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Abstract

The study of computational complexity has evolved significantly since the classification of
the Boolean satisfiability problem as NP-complete in 1971. With thousands of problems
now identified as NP-hard, understanding their tractability remains a central challenge,
particularly under the widely held assumption that P # NP. This thesis investigates the
parameterized complexity of several fundamental graph-theoretic problems, providing new

algorithmic insights and complexity classifications.

We analyze problems such as HARMLESS SET, DEFENSIVE ALLIANCES, OFFENSIVE AL-
LIANCES, LOCALLY & GLOBALLY MINIMAL ALLIANCES, and F-FREE EDGE DELETION,
as well as the maximum minimal versions of classical problems like MINIMUM-SEPARATOR
and ODD CYCLE TRANSVERSAL. Our research provides a comprehensive parameterized
complexity landscape for these problems by establishing their fixed-parameter tractability

(FPT) status, designing kernelization algorithms, and proving hardness results.

A key contribution of this thesis is the establishment of W|1]-hardness results for several
problems, including HARMLESS SET, DEFENSIVE ALLIANCE, and OFFENSIVE ALLIANCE,
when parameterized by restrictive structural parameters such as feedback vertex set number,
pathwidth, treedepth, and cluster vertex deletion number. Additionally, we develop fixed-
parameter tractable (FPT) algorithms for problems such as HARMLESS SET parameterized
by vertex integrity, neighborhood diversity, and twin cover, as well as for LOCALLY MINI-
MAL DEFENSIVE ALLIANCE when parameterized by solution size. In terms of kernelization
complexity, we show that DEFENSIVE ALLIANCE does not admit a polynomial kernel when
parameterized by vertex cover number, unless coNP C NP/poly, and we establish an XP
algorithm for this problem parameterized by clique-width. Moreover, we resolve open prob-
lems in the literature, such as proving the W][1]-hardness of 7,.;-FREE EDGE DELETION
parameterized by treewidth and determining the FPT classification of MAXIMUM MINI-
MAL st-SEPARATOR parameterized by solution size. Finally, we introduce new kernelization
techniques and subexponential algorithms for problems like LOCALLY MINIMAL DEFENSIVE
ALLIANCE on special graph classes, including planar graphs.

Beyond their theoretical significance, these results have potential applications in net-
work security, social influence analysis, and computational biology, where efficient structural

modifications play a crucial role. By leveraging advanced tools from graph theory, combina-

1X



torial optimization, and algorithm design, this thesis provides a refined perspective on the

tractability of NP-hard problems within the framework of parameterized complexity.
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Chapter 1

Introduction

In 1971, it was proved that the Boolean satisfiability problem is NP-complete. Later in
1972, Karp provided a list of 21 important NP-complete problems. To this date, thousands
of problems have been proved to be NP-hard. Unless the famous conjuncture P# NP fails, the
NP-hard problems do not admit a polynomial-time algorithm in the size of the input. Since
these problems cannot be solved efficiently in the general case, various frameworks have been
introduced to tackle them, such as Heuristic Algorithms, Approximation Algorithms, Exact
Exponential Algorithms, Randomized Algorithms etc. FEach of the above listed frameworks
compromise either in the quality of the solution or in the running time (or both). Downey
and Fellows introduced the framework of Parameterized Complexity in early 90’s for dealing
with hard problems. This framework measures the computational complexity of the problem
using the input size and an additional measurement which is called the parameter. The
most natural candidate for the parameter is the size of the solution we are looking for.
Although, in many cases we exploit the structural properties of the input instances such as
treewidth, vertex cover number, feedback vertex set number, clique width or solution quality,
among others. The overall goal is to identify interesting parameterizations of hard problems
where we can design algorithms running in time polynomial in the input size but possibly
exponential (or worse) in the small parameter. A problem is called fixed-parameter tractable
(FPT) if there exists an algorithm that solves it within a polynomial-time bound in the input
size, multiplied by a function of the parameter. Not all parameterized problems are fixed-
parameter tractable (FPT) under reasonable complexity-theoretic assumptions. There exists

a hierarchy of complexity classes, known as the W-hierarchy, which captures the hardness of



parameterized problems. Similar to NP-hardness, which provides evidence that a problem
is unlikely to be solvable in polynomial time, showing that a parameterized problem is hard
for one of the classes in the W-hierarchy provides evidence that the problem is unlikely to
be in FPT.

In this dissertation, we consider parameterized algorithms and complexity of the following
graph problems: harmless set, defensive and offensive alliances, locally and globally minimal
defensive alliances, F-free edge deletion, T, 1-free edge deletion, and maximum minimal

st-separation in graphs. We give below, section-wise, the problems considered.

1.1 Harmless Set

Social networks are used not only to stay in touch with friends and family, but also to
spread and receive information on specific products and services. Much of human interac-
tion happens on a local level, wherein our opinions and actions are affected most by our
peers, families, and neighbours. The spread of information, propagated through such social
interactions, is a well-documented and well-studied topic. Kempe, Kleinberg, and Tardos
[89] initiated a model to study the spread of influence through a social network. One of the
most well-known problems that appear in this context is TARGET SET SELECTION intro-
duced by Chen [26] and defined as follows. We are given a graph, modelling a social network,
where each node v has a (fixed) threshold ¢(v). The node will adopt a new product if at
least t(v) of its neighbours adopt it. Their goal is to find a small set S of nodes such that
targeting the product to S would lead to the adoption of the product by a large number of
nodes in the graph. This problem may occur for example in the context of disease propa-
gation, viral marketing or even faults in distributed computing [39, [112]. For example, to
explain disease propagation, consider a network in which each person is in one of two states:
infected or uninfected. Suppose a person v becomes infected if at least ¢(v) of its neighbours
are infected. We also assume that a person is never cured once infected. Here a target set is
a set of people such that if they are infected at the beginning, then eventually everyone in
the network becomes infected. According to Dreyer and Roberts [39] “Such a set of people
represents a set of individuals a bioterrorist could infect so as to be sure to infect every-
one eventually. Defense against infection would then amount to finding good vaccination or

quarantine strategies." TARGET SET SELECTION has received considerable attention in a



series of papers from classical complexity [39] 22| 27, [114], polynomial time approximability
[26, [1], parameterized approximability [I1], and parameterized complexity [14] 29 [111]. A
natural research direction considering this fact is to look for the complexity of variants or

constrained versions of this problem.

Consider the following variant of the marketing problem mentioned above: As before, we
are given a graph that represents social interactions, and each node v has a threshold t(v).
A person buys the product if it is recommended by at least t(v) people. However, social
interactions can spread negative reviews as well, and so, experimenting with a novel product
can also harm the company’s reputation if the product is not well received by the target set.
In such a scenario, it is beneficial for the company to approach the largest group of people,
whose possibly negative reviews do not influence other members of society. This motivates
the idea of a harmless set in a graph. Indeed, the company can approach a target set for

marketing purposes post a successful experiment phase.

The notion of a harmless set was introduced by Bazgan and Chopin [10]. Roughly
speaking, a harmless set is a subset S of nodes, such that every node of the graph has
fewer than t(v) neighbours in the set S. By definition, a harmless set cannot initiate a
cascading behaviour in a network, and in some sense is a converse notion of a target set.
The harmlessness of the set can be emphasised by noting that the problem searches for
isolated vertices when all thresholds are set to 1. Large harmless sets can be used as a strong
first line of defence while mitigating disasters, or to maximise profitable actions without
spreading adverse effects. Consider the following instances. Suppose a company has network
towers in each area of a city. The residents of each area use a combination of surrounding
towers to receive network and would switch to some other company if a certain number of
towers stop working. If the company wishes to cut costs by removing some towers without
losing any customers, then it must look for a maximum harmless set in the graph with towers
as nodes and whose edges connect geographically neighbouring towers. Similarly, a harmless
set could also be useful to find trees in a forest for conducting experiments, such that the
experiment can be done in natural conditions without affecting any other trees. We now
define these notions more formally. A harmless set consists of a set S of vertices with the

property that no propagation occurs if any subset of S gets activated.

Definition 1.1.1. [10] Let G = (V, E) be an undirected graph, and ¢ : V' — N a threshold

3



function. A subset S C V is a harmless set of G, if for every vertex v € V' we have |N(v) N
S| < t(v).

Note that in the definition of a harmless set, the threshold condition is imposed on every
vertex, including those in the solution set S. According to Bazgan and Chopin [10], “Another
perhaps more natural definition could have been a set S such that every vertex v ¢ S has
fewer than ¢(v) neighbours in S. This definition raises the following two problems. First, it
makes HARMLESS SET meaningless as a trivial solution would be to take the whole set of
vertices of the input graph. Second, there might be some propagation steps inside S if some
vertices are activated in it." Here, we consider HARMLESS SET under structural parameters.

We define the problem as follows:

HARMLESS SET
Input: A graph G = (V, E), a threshold function ¢ : V' — N where 1 < t(v) < d(v) for

every v € V', and an integer k.

Question: Is there a harmless set S C V of size at least k7

If the threshold function is defined by t(v) = [@} for all v € V then we call the problem
the MAJORITY HARMLESS SET problem. Bazgan and Chopin [10] introduced this problem.
We show that the problem is W|[1|-hard when parameterized by several restrictive structural
parameters, including the feedback vertex set number, pathwidth, treedepth, and cluster
vertex deletion number of the input graph. On the positive side, we present fixed-parameter
tractable (FPT) algorithms when the problem is parameterized by vertex integrity, neighbor-
hood diversity, or twin cover. Additionally, we establish an upper bound on the complexity

by providing an XP algorithm parameterized by clique-width.

1.2 Defensive and Offensive Alliances

In real life, an alliance is a collection of people, groups, or states such that the union is
stronger than individual. The alliance can be either to achieve some common purpose, to
protect against attack, or to assert collective will against others. This motivates the defini-
tions of defensive alliances in graphs. The properties of alliances in graphs were first studied

by Kristiansen, Hedetniemi, and Hedetniemi [95]. They introduced defensive, offensive and
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powerful alliances. An alliance is global if it is a dominating set. The alliance problems have
been studied extensively during last fifteen years [56] [120] 25 [117] [121], and generalizations

called r-alliances are also studied [119].

Definition 1.2.1. A non-empty set S C V is a defensive alliance in G = (V, E) if dg(v)+1 >
dse(v) for all v € S.

We often use the terms defenders and attackers of an element v of a defensive alliance S.
By these we mean the sets N[v] NS and N[v]\ S, respectively. A vertex v € S is said to be
protected if the number of defenders of v is greater than or equal to the number of attackers
of v, that is, |[IN[v]NS| = dg(v)+1 > dge(v) = [N[v]\ S|. A set S CV is a defensive alliance
if every vertex in S is protected. In this thesis, we consider DEFENSIVE ALLIANCE under

structural parameters. We define the problem as follows:

DEFENSIVE ALLIANCE
Input: An undirected graph G = (V, E') and an integer k£ > 1.
Question: Is there a defensive alliance S C V(G) such that |S| < k?

We study the parameterized complexity of DEFENSIVE ALLIANCE, where the aim is to
find a minimum size defensive alliance. Our main results are the following: (1) DEFEN-
SIVE ALLIANCE has been studied extensively during the last twenty years, but the question
whether it is FPT when parameterized by feedback vertex set has still remained open. We
prove that the problem is W][l]-hard parameterized by a wide range of fairly restrictive
structural parameters such as the feedback vertex set number, treewidth, pathwidth, and
treedepth of the input graph; (2) the problem parameterized by the vertex cover number of
the input graph does not admit a polynomial compression unless coNP C NP /poly, (3) it
does not admit 2° algorithm under ETH, and (4) DEFENSIVE ALLIANCE on circle graphs
is NP-complete.

Definition 1.2.2. A non-empty set S C V' is an offensive alliance in G if dg(v) > dge(v)+1
for all v € N(9).

Since each vertex in N(S) has more neighbors in S than in S, we say that every vertex in
N(S) is vulnerable to possible attack by vertices in S. Equivalently, since an attack by the
vertices in S on the vertices in V'\ S can result in no worse than a “tie” for S, we say that
S can effectively attack N(S).



Definition 1.2.3. A non-empty set S C V' is a strong offensive alliance in G if dg(v) >
dse(v) + 2 for all v € N(S5).

We consider OFFENSIVE ALLIANCE and STRONG OFFENSIVE ALLIANCE problems under

structural parameters. We define these problems as follows:

OFFENSIVE ALLIANCE
Input: An undirected graph G = (V, E) and an integer r > 1.
Question: Is there an offensive alliance S C V(G) such that |S| < r?

STRONG OFFENSIVE ALLIANCE
Input: An undirected graph G = (V, E') and an integer r > 1.
Question: Is there a strong offensive alliance S C V(G) such that |S| < r?

Alliances have been used to study problems such as classification and clustering problems,
understanding communities on the internet, protocols for distribution etc. [122] [I18]. The
data clustering problem relies on the concept of partitioning the vertices of the graph into

multiple strong defensive alliances.

The goal here is to provide new insight into the complexity of OFFENSIVE ALLIANCE
parameterized by the structure of the input graph. We resolve the problem with most of
the structural parameters. We mostly discuss the parameters that deal with sparseness of
graph. We show that OFFENSIVE ALLIANCE is W|[1]-hard parameterized by any of the
following parameters: the feedback vertex set number, treewidth, pathwidth, and treedepth
of the input graph. Interestingly, our result is significantly stronger since we show that the
problem is W[1]-hard parameterized by the size of a vertex deletion set into trees of height
at most seven. Next, we turn our attention to parameters the vertex cover number of the
input graph and the solution size. It is known that the problem admits FPT algorithms
parameterized by each of these parameters individually. As there is no hope to get FPT
algorithms with small structural parameters, we need to make the most out of these two
parameters by obtaining efficient algorithms and kernels. The FPT algorithm mentioned
in [43], has a running time O*(2°("°87)) where 7 is the solution size. The first question
that arises from here is whether we can get a single exponential algorithm? We answer this
question in a negative way by proving that unless ETH fails, OFFENSIVE ALLIANCE cannot
be solved in time O*(2°"°¢7)). For the parameter vertex cover number, the FPT algorithm

O(2vc(G))

mentioned in [92] has running time O*((27¢(®)) ) where vc(G) is the vertex cover number



of the input graph. In this case, we improve the running time to O* (VC(G)O(VC(G)). Finally,
we show that it is unlikely to get a polynomial kernel when parameterized by both of these

parameters combined.

In search of efficient algorithms, alliance problems have been studied on special graph
classes. There are polynomial time algorithms for finding minimum alliances in trees [23]
84]. A polynomial time algorithm for finding minimum defensive alliance in series parallel
graph is given in [83]. But still, alliance problems remained unexplored on special classes of
intersection graphs such as interval graphs, circle graphs, circular arc graphs, unit disk graphs
etc. We show that the problem remains NP-hard even when restricted to bipartite, chordal,
split and circle graphs. We also prove that the known algorithms on general graphs and
apex graphs are unlikely to improve. This is done by showing that OFFENSIVE ALLIANCE
cannot be solved in 2° time even when restricted to bipartite graphs and also it cannot be

solved in 2°0V™ time even when restricted to apex graphs, unless ETH fails.

1.3 Locally and Globally Minimal Defensive Alliances

Throughout history, humans have formed communities, guilds, faiths, etc in the hope of
coming together with a group of people having similar requirements, visions, and goals.
Their reasons to do so usually rest on the fact that any group with common interests often
provides added mutual benefits to the union in fields of trade, culture, defense, etc, as
compared to the individual. Such activities are commonly seen today in geopolitics, cultures,
trades, economics, unions, etc. and are popularly termed as alliances. Based on an alliance
structure, formation, and goals, many variations of the problem exist in graph theory. A
defensive alliance is usually formed to defends its members against non-members; hence, it is
natural to ask that each member of the alliance should have more friends within the alliance
(including oneself) than outside. These lead to algorithmic problems in the formation of
alliances. The main focus here is the study of one such problem from the perspective of

parameterized complexity.

A non-empty set D of vertices of a graph is a defensive alliance if, for each element of D,
the majority of its neighbors are in D. In the DEFENSIVE ALLIANCE problem, given a graph
GG and a positive integer k, the objective is to decide whether there exists a vertex subset

of size at most k, that forms a defensive alliance. During the last 20 years, the DEFENSIVE
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ALLIANCE problem, and several of its variants, have been studied extensively from both the
combinatorial and computational perspective |95 113, [12] 21] 120, 117 119, 1211 [43] [16]
491 [52] 84], (561 21] [51]. As expected, the problems of finding small defensive and offensive

alliances are NP-complete.

The focus has chiefly been on finding small alliances, although studying large alliances
makes a lot of sense given the original motivation behind these notions, and they were
actually also outlined in the very first papers on alliances [95]. To remedy this situation,
researchers have considered the notion of minimal defensive alliance problems. We first
caution that being a defensive alliance is not a hereditary property, that is, a superset or
subset of a defensive alliance is not necessarily a defensive alliance. Shafique [82] called an
alliance a locally minimal alliance if the set obtained by removing any vertex of the alliance
is not an alliance. Bazgan et al. [12] considered another notion of alliance that they called a
globally minimal alliance, which has the property that no proper subset is an alliance. Bazgan
et al. [12] proved that deciding if a graph contains a locally minimal defensive alliance of size
at least k is NP-complete, even when restricted to bipartite graphs with an average degree
less than 5.6. This naturally leads to the question, what is the tractability of this problem
when considered from the algorithmic paradigms meant to cope with NP-hardness, such as

parameterized complexity?

Here, we take up such a study and consider the LOCALLY MINIMAL DEFENSIVE AL-
LIANCE problem from the perspective of parameterized complexity. In this problem, given
an undirected graph G = (V| F) and an integer k € N, the goal is to check whether G has a
locally minimal defensive alliance of size at least k, say D. Such a vertex set D is called a
solution. The parameterized complexity of alliance problems is well-studied with both nat-
ural and structural parameters. The problem of finding a defensive alliance of size at most
k admits an FPT algorithm when parameterized by solution size, see [50]. We complement
this positive result by showing that LOCALLY MINIMAL DEFENSIVE ALLIANCE also admits
an FPT algorithm, which uses a similar approach of extreme combinatorics mentioned in
[48].

A vertex v € D is said to be protected if degp,(v) + 1 > degp.(v). Here v has degp(v) + 1
defenders and degp.(v) attackers in G. A set D C V is a defensive alliance if every vertex

in D is protected.

Definition 1.3.1. A vertex v € D is said to be marginally protected if it becomes unprotected



when any of its neighbors in D is moved from D to V' \ D. A vertex v € D is said to be
overprotected if it remains protected even when any of its neighbors is moved from D to
V\ D.

Definition 1.3.2. [12] A defensive alliance D is called a locally minimal defensive alliance

if for any v € D, D\ {v} is not a defensive alliance.

It is important to note that if D is a locally minimal defensive alliance, then for every vertex

v € D, at least one of its neighbors in D is marginally protected.

Definition 1.3.3. [12] A defensive alliance D is a globally minimal defensive alliance or

shorter minimal alliance if no proper subset is a defensive alliance.

()

@‘8{1/4‘@%\@\@

Figure 1.1: The set D; = {2,3,5,6,8,9} is a locally minimal defensive alliance of size 6 and
Dy ={1,2,4,6,8} is a globally minimal defensive alliance of size 5 in G.

It may be noted that every globally minimal defensive alliance is also a locally minimal
defensive alliance but the converse is not true. A defensive alliance D is connected if the
subgraph induced by D is connected. An alliance D is called a connected locally minimal
alliance if for any v € D, D \ {v} is not an alliance and the graph induced by vertices in
D is a connected graph. Notice that any globally minimal alliance is always connected. We
consider LOCALLY MINIMAL DEFENSIVE ALLIANCE. We define the problem as follows:

LocALLY MINIMAL DEFENSIVE ALLIANCE
Input: An undirected graph G = (V, E)) and an integer k.

Question: Does G have a locally minimal defensive alliance D with |D| > k7

The parameterized complexity of alliance problems is well-studied with both natural
and structural parameters. The problem of finding a defensive alliance of size at most &
admits an FPT algorithm when parameterized by solution size, see [50|. We complement
this positive result by showing that LOCALLY MINIMAL DEFENSIVE ALLIANCE also admits
an FPT algorithm, which uses a similar approach of extreme combinatorics mentioned in
[48]. Our main contribution is that LOCALLY MINIMAL DEFENSIVE ALLIANCE is FPT when

parameterized by solution size.



In this thesis, we also consider GLOBALLY MINIMAL DEFENSIVE ALLIANCE under various
structural parameters. In the literature, a defensive alliance S is called a global defensive
alliance if S is a dominating set. It is important to note that the globally minimal defensive

alliance problem is different from the global defensive alliance problem.

Observation 1.3.1. Let S be a globally minimal defensive alliance of size at least two in

G. Then S can never contain a vertex of degree one.

This can be proved by contradiction. Suppose S contains a vertex v of degree one. Note
that {v} is a proper subset of S and it is a defensive alliance, a contradiction to the fact

that S is a globally minimal defensive alliance.

A defensive alliance S is connected if the subgraph G[S] induced by S is connected. Notice

that any globally minimal defensive alliance is always connected.
Observation 1.3.2. If a non-empty set S C V' is connected and each v € S is marginally

protected, then S is a globally minimal defensive alliance.

Note that although the conditions of this observation are sufficient to assure that S is a
globally minimal defensive alliance, they are certainly not necessary. For example, consider
the graph G shown in Figure Note that S = {z,y, u1,us} is a globally minimal defensive

alliance, but u; and uy are not marginally protected.

Uy U2 us Uy Us

2 Y

Figure 1.2: S = {x,y,us,us} is a globally minimal defensive alliance in G.

We define the problem as follows:

GLOBALLY MINIMAL DEFENSIVE ALLIANCE
Input: An undirected graph G = (V, E') and an integer k > 2.
Question: Is there a globally minimal defensive alliance S C V(G) such that |S| > k?
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We provide new insights into the complexity of GLOBALLY MINIMAL DEFENSIVE AL-
LIANCE parameterized by the structure of the input graph. We show that the problem is
FPT parameterized by the neighbourhood diversity of the input graph. This result implies
that the problem is also FPT when parameterized by the vertex cover number. We prove
that the problem, parameterized by the vertex cover number of the input graph, does not
admit a polynomial compression unless coNP C NP /poly. We show that the problem is
W][1]-hard parameterized by a wide range of fairly restrictive structural parameters such as
the feedback vertex set number, pathwidth, treewidth and treedepth. We also prove that,
given a vertex r € V(G), deciding if G has a globally minimal defensive alliance of any size

containing vertex r is NP-complete.

1.4 F-Free Edge Deletion

Animal diseases pose a risk to public health and cause damage to businesses and the economy
at large. Among different reasons for livestock disease, livestock movements constitute major
routes for the spread of an infectious livestock disease |[76|. For example, the long-range
movement of sheep in combination with local transmission resulted in the FMD epidemic
in the UK in 2001 [76} [106]. Livestock movements could, therefore, provide insight into
the structure of the underlying transmission network and thus allow early detection and
more effective management of infectious diseases [90]. To do this, mathematical modelling
has been employed widely to describe contact patterns of livestock movements and analyse
their potential use for designing disease control strategies [90]. For the purpose of modelling
disease spread among farm animals, it is common to consider a transmission network with

farms as nodes and livestock movement between farms as edges.

In order to control or limit the spread of a disease on this sort of transmission network,
we focus our attention on edge deletion, which might correspond to forbidden trade partners
or more reasonably, extra vaccinations or disease surveillance along certain trade routes.
Introducing extra control of this kind is costly, so it is important to ensure that this is
done as efficiently as possible. Many properties that might be desirable from the point of
view of restricting the spread of a disease can be expressed in terms of forbidden subgraphs:
delete edges so that each connected component in the resulting graph has at most h vertices,

is equivalent to edge-deletion to a graph avoiding all trees on h + 1 vertices. In general,
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given a graph G = (V, E) and a set F of forbidden subgraphs, we study the F-FREE EDGE
DELETION problem, where the goal is to remove a minimum number of edges such that the
resulting graph does not contain any F' € F as a (not necessarily induced) subgraph. We

define the problem as follows:

F-FREE EDGE DELETION

Input: A graph G = (V| E), a set F of forbidden subgraphs and a positive integer k.
Question: Does there exist £’ C F(G) with |E’| = k such that G\ £’ does not contain
any F' € F as a (not necessarily induced) subgraph?

Enright and Meeks (Algorithmica, 2018) gave an algorithm to solve F-FREE EDGE
DELETION whose running time on an n-vertex graph G of treewidth tw(G) is bounded by
20(F1w(&)"), if every graph in F has at most r vertices. We complement this result by
showing that F-FREE EDGE DELETION is W][1]-hard when parameterized by tw(G) + | F]|.
We also show that F-FREE EDGE DELETION is W|2|-hard when parameterized by the
combined parameters solution size, the feedback vertex set number and pathwidth of the

input graph.

1.5  7T,.1-Free Edge Deletion

Graph theoretic problems show immense applicability in real-life scenarios such as those
involving transportation, flows, relationships and complex systems. The spread of a disease,
for instance, can be modeled via such mathematical verbiage. A way to describe this idea
is as follows: Livestock are often carriers of various pathogens, and thus their movement,
among other reasons, plays a key role in inducing a pandemic amongst humans or livestock
[76]. To point at a particular instance, the cause of the 2001 FMD epidemic in the UK can
be traced to long-range movements of sheep combined with local transmissions amongst the
flocks [76] [106]. Thus, an effective way of mitigating the spread of livestock diseases could
be modelled by studying the underlying transmission networks based on the routes of cattle
movement. Such models can allow for early detection and better management of disease

control strategies [90].

More precisely, we consider a graph with its nodes as livestock farms and edges denoting

common routes of livestock movement between farms. Using this graph, we can identify
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certain edges, or routes, such that connected components of the network obtained by deleting
these edges are manageably small in size. Then, these deleted edges will precisely correspond
to those trade routes which require more disease surveillance, vaccination stops, movement
controls etc, required for disease management. In essence, we have divided our disease
control strategies to a few routes and smaller manageable networks. Naturally, for maximum
efficiency, one would also like to minimise the number of edges being deleted, and this
provides us enough information to chalk out a graph theoretic problem, which we shall
describe in the next section. It should be noted that the damaging effect such pandemics
have on public health, economies, and businesses, essentially validates the need for such a

study.

Many properties that might be desirable from the point of view of restricting the spread
of a disease can be expressed in terms of forbidden subgraphs: delete edges so that each
connected component in the resulting graph has at most h vertices, is equivalent to edge-
deletion to a graph avoiding all trees on h + 1 vertices. One question of particular relevance
to epidemiology would be the complexity of the problem on planar graphs; this would be rel-
evant for considering the spread of a disease based on the geographic location (in situations
where a disease is likely to be transmitted between animals in adjacent fields) [44]. Further-
more, in practice animal movement networks can capture more information when considered
as directed graphs. The natural generalisation of 7, ;-FREE EDGE DELETION to directed
graphs in this contexts follows: given a directed graph and a positive integer k, the goal is
to verify whether it is possible to delete at most k edges from a given directed graph so that
the maximum number of vertices reachable from any given starting vertex is at most h. The
Trhi1-FREE EDGE DELETION problem on planar graph and directed graphs were introduced
by Enright and Meeks [44]. Exploiting information on the direction of movements might
allow more efficient algorithms for 7;,,-FREE EDGE DELETION; a natural first question
would be to consider whether there exists an efficient algorithm to solve this problem on

directed acyclic graphs.

We are interested in solving the 7, 1-FREE EDGE DELETION problem. This problem is
of particular interest because it can be seen as the problem of removing connections so as
to obtain a network where each connected component has at most h vertices, an abstract
view of numerous real world problems. Here, we study 7,,1-FREE EDGE DELETION and
Trn+1-FREE ARC DELETION. We define the problems as follows:
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Thi1-FREE EDGE DELETION

Input: An undirected graph G = (V, E), and two positive integers k and h.

Question: Does there exist E' C E(G) with |E’| < k such that each connected compo-
nent in G\ £’ has at most h vertices, that is, the graph G \ E’ does not contain any tree

on h + 1 vertices as a (not necessarily induced) subgraph?

A natural generalization of this problem to directed graphs in this context would be to con-
sider whether it is possible to delete at most k£ arcs from a given directed graph so that the

maximum number of vertices reachable from any given starting vertex is at most h.

Thi1-FREE ARC DELETION
Input: A directed graph G = (V, E), and two positive integers k and h.
Question: Does there exist E' C F(G) with |E’| < k such that the maximum number

of vertices reachable from any given starting vertex is at most h in G \ E'?

Enright and Meeks (Algorithmica, 2018) gave an algorithm to solve 7,,1-FREE EDGE
DELETION whose running time on an n-vertex graph G of treewidth tw(G) is bounded by
O((tw(G)h)**(@)n). We complement this result by showing that 7,,1-FREE EDGE DELE-
TION is W][1]-hard when parameterized by tw(G) alone. We thereby resolve a conjecture
stated by Enright and Meeks (Algorithmica, 2018) concerning the complexity of 7;,.;-FREE
EDGE DELETION parameterized by the treewidth of the input graph. One question of par-
ticular relevance to epidemiology would be the complexity of the problem on planar graphs;
this would be relevant for considering the spread of a disease based on the geographic loca-
tion. We prove that the 7,,1-FREE EDGE DELETION problem is NP-complete even when
restricted to planar graphs. We also show that the 7,,1-FREE EDGE DELETION problem is

W|2|-hard when parameterized by the solution size on directed acyclic graphs.

1.6 MaxMin Separation Problems

We study fixed-parameter tractability of two fundamental MaxMin separation problems:
MAXIMUM MINIMUM st-SEPARATOR (MAXMIN st-SEP) and MAXIMUM MINIMUM ODD
CYCLE TRANSVERSAL (MAXMIN OCT). In the MAXMIN st-SEP problem, the input is an

undirected graph G, two distinct vertices s,t of G and a positive integer k. The goal is to
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determine if there exists a subset of vertices Z, of size at least k, such that Z is a minimal
st-separator in GG. That is, the deletion of Z disconnects s and ¢ and the deletion of any
proper subset of Z results in a graph where s and t are connected. Similarly, in the MAXMIN
OCT problem, given G, k, the goal is to determine if there exists a set of vertices Z, of size
at least k, such that Z intersects all odd length cycles in G and Z is minimal, that is no

proper subset of Z intersects all odd length cycles in G.

In contrast to the classical polynomial-time solvable st-SEPARATOR problem, where the
goal is to find an st-separator of size at most k, the MAXMIN st-SEP problem is NP-hard [81].
The MAXMIN OCT problem can also be shown to be NP-hard by giving a reduction from
the MAXMIN st-SEP problem.

MAXMIN versions of several classical vertex/edge deletion minimization problems have
been studied in the literature. The original motivation behind studying such versions is that
the size of the solution of the MAXMIN versions reflects on the worst-case guarantees of a
greedy heuristic. In addition to this, the MAXMIN problems have received a lot of attention
also because of their deep combinatorial structure which makes them stubborn even towards
basic algorithmic ideas. As we will highlight later, neither greedy, nor an exhaustive-search
strategy like branching, works for the MAXMIN versions of even the “simplest” problems with-
out significant effort. The MAXMIN versions are even harder to approximate. For example,
the classic VERTEX COVER and FEEDBACK VERTEX SET problems admit 2-approximation
algorithms [5], which are tight under the Unique Games Conjecture [91]. But the MAXMIN
VERTEX COVER admits a n'/2-approximation, which is tight unless P = NP [19] [130], and
the MAXMIN FEEDBACK VERTEX SET admits a tight n?/3-approximation [41].

We note here that the study of MaxMin versions of classical deletion problems is not the
only proposed way of understanding worst-case heursitics guarantees (though in this work
we only focus on such versions). Several variations of different problems have been defined
whose core is similar. This includes problems like b-COLORING, GRUNDY COLORING etc,
which are analogs for the classic CHROMATIC NUMBER problem.

We demonstrate that MAXMIN versions of two fundamental separation problems: MAX-
IMUM MINIMAL st-SEPARATOR and MAXIMUM MINIMAL ODD CYCLE TRANSVERSAL
(OCT) problems are fixed-parameter tractable parameterized by k. Our FPT algorithm
for MAXIMUM MINIMAL st-SEPARATOR answers the open question by Hanaka, Bodlaender,
van der Zanden & Ono [TCS 2019].
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1.7 Literature Survey

In this section, we review the existing literature on the specific problems studied in this

dissertation.

Harmless Set: Given a graph G = (V, E), a threshold function ¢ : V — N and an
integer k, we study HARMLESS SET, where the goal is to find a subset of vertices S C
V' of size at least k such that every vertex v € V has fewer than t(v) neighbours in S.
Bazgan and Chopin [10] studied the parameterized complexity of HARMLESS SET and the
approximation of the associated maximization problem. When the parameter is k, they
proved that HARMLESS SET is W|2]|-complete in general and W][1]-complete if all thresholds
are bounded by a constant. When each threshold is equal to the degree of the vertex,
they showed that HARMLESS SET is fixed-parameter tractable parameterized by k£ and the
maximization version is APX-complete. They gave a polynomial-time approximation scheme
for planar graphs. Drange, Muzi and Reidl |38] showed that HARMLESS SET parameterized
by k is fixed-parameter tractable in nowhere dense classes. Bazgan and Chopin [10] gave an
algorithm to solve HARMLESS SET whose running time on an n-vertex graph G of treewidth
tw(G) is bounded by O(kK°®#(&) . n) where k is the solution size and we are provided a tree
decomposition of width tw(G) as part of the input. However, it remains open whether the
problem might belong to FPT when parameterized only by the treewidth tw(G). In this
thesis, we resolve this open problem by showing that HARMLESS SET is indeed W|[1]|-hard
when parameterized by tw(G) alone, even when restricted to bipartite graphs. Drange, Muzi
and Reidl [38] proved that HARMLESS SET is W|[1|-hard when parameterized by a modulator
to a 2-spider-forest. That is, they independently proved that the problem is W|1|-hard for
parameters like treewidth, pathwidth, and even treedepth. They also proved that HARMLESS
SET is fixed-parameter tractable when parameterized by the vertex cover number of the input
graph and the problem is solvable in time O(2°) . n) on apex-minor-free graphs. Gaikwad
and Maity [65] showed that MAJORITY HARMLESS SET is W|[1|-hard when parameterized
by the treewidth of the input graph. Bazgan and Chopin [10] introduced the parametric
dual problem (n — k)-HARMLESS SET which asks for the existence of a harmless set of size
at least n — k. Here n is the number of vertices in the input graph, and the parameter is k.
They showed that the parametric dual problem (n — k)-HARMLESS SET is fixed-parameter
tractable for a large family of threshold functions. Bazgan and Chopin [10] also proved that

finding a maximum harmless set can be done in O(log(tmax) - n) time for trees and finally
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they showed that given a tree decomposition of width tw(G) of G, a maximum harmless set
can be computed in time tﬁg‘;‘”(c)) -n where .« 1S the maximum threshold. Gaikwad and
Maity [68] [65] provided new insights into the complexity of the HARMLESS SET problem
parameterized by the structure of the input graph. They presented several results, both
tractability and intractability, as follows: (i) HARMLESS SET is FPT when parameterized
by any of the following parameters: the neighbourhood diversity, twin cover, and vertex
integrity of the input graph. These results are obtained via Integer Linear Programming
(ILP). (ii) MAJORITY HARMLESS SET is W[1]-hard when parameterized by the treewidth
of the input graph. (iii) HARMLESS SET is W|[1]-hard when parameterized by the size of a
vertex deletion set into trees of height at most 3, even when restricted to bipartite graphs.
A similar result is shown independently by Drange, Muzi and Reidl [38]. (iv) HARMLESS
SET is W|[1]-hard when parameterized by cluster vertex deletion set. (v) Given a graph
G and an irredundant c-expression of G, HARMLESS SET can be solved in XP-time when

parameterized by clique-width.

Defensive and Offensive Alliances: The decision version for several types of alliances
have been shown to be NP-complete. For an integer r, a nonempty set S C V(G) is a
defensive r-alliance if for each v € S, I[IN(v) N S| > |[N(v) \ S| + 7. A set is a defensive
alliance if it is a defensive (—1)-alliance. A defensive r-alliance S is global if S is a dominating
set. The defensive r-alliance problem is NP-complete for any r [119]. The defensive alliance
problem is NP-complete even when restricted to split, chordal and bipartite graph [84].
There are polynomial time algorithms for finding minimum alliances in trees [23] [84]. A
polynomial time algorithm for finding minimum defensive alliance in series parallel graph
is presented in [83]. There has also been some work on the parameterized complexity of
alliance problems. It is known that DEFENSIVE ALLIANCE and OFFENSIVE ALLIANCE
are fixed-parameter tractable when parameterized by the solution size [43] 50]. Alliance
problems have been studied with respect to structural graph parameters. In this paper,
we show that the problems for all variants are efficiently solvable for much larger graph
classes. Kiyomia and Otachi [92] proved that alliance problems can be solved in polynomial
time for graphs of bounded clique-width. They also showed that the problems are fixed-
parameter tractable when parameterized by the vertex cover number. Ensico [43] showed
that the problems of finding minimum size defensive alliances and global defensive alliances
are fixed-parameter tractable when parameterized by the combined parameters treewidth
and maximum degree. Treewidth [116}[17] is one of the most extensively studied structural

parameters in parameterized complexity. It indicates how close a graph is to being a tree.
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It is particularly interesting because there are many hard problems which become tractable
on instances of bounded treewidth. It has also been observed that the problem instances for
several practical applications exhibit small treewidth [17,[125]. Hence it is very appealing to
obtain FPT algorithms for the DEFENSIVE ALLIANCE and OFFENSIVE ALLIANCE problems
using this parameter. Bliem and Woltran [16] proved that defensive alliance problem is
W|[1]-hard when parameterized by the treewidth of the input graph. Gaikwad and Maity
|60, [67] proved that the OFFENSIVE ALLIANCE problem is W][1]-hard when parameterized
by the treewidth of the input graph. This puts these two problems among the few problems
that are FPT when parameterized by solution size but not when parameterized by treewidth
(unless FPT=W][1]).

Locally and Globally Minimal Defensive Alliances: Bazgan, Fernau, and Tuza [12]
demonstrated that determining whether a graph contains a locally minimal strong defensive
alliance of size at least k is NP-complete, even when restricted to bipartite graphs with an av-
erage degree of less than 3.6. Similarly, they showed that deciding whether a graph contains
a locally minimal defensive alliance of size at least k remains NP-complete under the same
restrictions, with an average degree of less than 5.6. Furthermore, the authors proved that
determining whether a graph contains a connected locally minimal strong defensive alliance
or a connected locally minimal defensive alliance of size at least k is NP-complete, even for
bipartite graphs with an average degree of less than 2 + €, for any € > 0. Bazgan et al. [12]
proved that deciding if a graph contains a globally minimal strong defensive alliance of size at
least k is NP-complete, even for cubic graphs. Moreover, deciding if a graph contains a glob-
ally minimal defensive alliance of size at least k is NP-complete, even for graphs of degree 3
or 4 [12]. Gaikwad, Maity and Tripathi [72] provided new insights into the complexity of the
LocALLY MINIMAL DEFENSIVE ALLIANCE problem parameterized by the structure of the
input graph. They presented several results, both tractability and intractability, as follows:
(i) LocALLY MINIMAL DEFENSIVE ALLIANCE is NP-complete, even when restricted to pla-
nar graphs, (ii) it is fixed-parameter tractable (FPT) when parametrized by neighbourhood
diversity, (iii) the problem parameterized by treewidth is W[1]-hard and thus not FPT (un-
less FPT = W|1]), (iv) it can be solved in polynomial time for graphs of bounded treewidth.
Gaikwad and Maity [63] gave a polynomial-time algorithm to find a maximum size globally
minimal defensive alliance when the input graph happens to be a tree. They also proved
that GLOBALLY MINIMAL DEFENSIVE ALLIANCE is W|[1]-hard when parameterized by the
treewidth of the input graph. Gaikwad, Maity and Saurabh [69] showed that the LOCALLY
MINIMAL DEFENSIVE ALLIANCE problem is FPT when parameterized by solution size. For
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graphs that are Cs-free and Cy-free with minimum degree at least 2, they provided a kernel of
size k). When restricted to planar graphs with minimum degree at least 2, they designed
an algorithm with running time kOWR RO,

F-Free Edge Deletion: If 7 is a property on graphs or digraphs, the edge-deletion prob-
lem can be stated as follows: find the minimum number of edges whose deletion results in
a subgraph (or subdigraph) satisfying property m. Several well-studied graph problems can
be formulated as edge-deletion problems. Yannakakis [128] showed that the edge-deletion
problem is NP-complete for the following properties: (1) without cycles of specified length
¢, for any fixed ¢ > 3, (2) connected and degree-constrained, (3) outer-planar, (4) tran-
sitive digraph, (5) line-invertible, (6) bipartite, (7) transitively orientable. Watanabe, Ae,
and Nakamra [126] showed that the edge-deletion problem is NP-complete if 7 is finitely
characterizable by 3-connected graphs. Natanzon, Shamir and Sharan [109] proved the NP-
hardness of edge-deletion problems with respect to some well-studied classes of graphs. These
include perfect, chordal, chain, comparability, split and asteroidal triple free graphs. This
problem has also been studied in generality under paradigms like approximation |57} [105]
and parameterized complexity |20, [79]. Cai [20] showed that edge-deletion to a graph class
characterisable by a finite set of forbidden induced subgraphs is fixed-parameter tractable
when parameterized by k (the number of edges to delete); he gave an algorithm to solve
the problem in time O(r% - n"*1), where n is the number of vertices in the input graph and
r is the maximum number of vertices in a forbidden induced subgraph. FPT algorithms
have been obtained for the problem of determining whether there are k edges whose deletion
results in a split graph [75] and to chain, split, threshold, and co-trivially perfect graphs [79].
Enright and Meeks [44] gave an algorithm for the F-FREE EDGE DELETION problem with
running time 2007 ™))y where tw(G) is the treewidth of the input graph G and r is the
maximum number of vertices in any element of F. This is a significant improvement on Cai’s
algorithm [20] but does not lead to a practical algorithm for addressing real world problems.
Gaikwad and Maity [62] complemented this result by showing that F-FREE EDGE DELE-
TION is W][1]-hard when parameterized by tw(G) + |F|. They also showed that F-FREE
EDGE DELETION is W|2]-hard when parameterized by the combined parameters: solution
size, the feedback vertex set number and pathwidth of the input graph.

Trni1-Free Edge Deletion: The special case of F-FREE EDGE DELETION problem in which
F is the set of all trees on h+ 1 vertices is of particular interest from the point of view of the

control of disease in livestock. Enright and Meeks [44] have proved that 7,,,-FREE EDGE
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DELETION is NP-hard for every h > 3. Enright and Meeks [44] have derived an improved
algorithm for this special case, running in time O((tw(G)h)**(@n). However, it remained
open whether the problem might belong to FPT when parameterized only by the treewidth;
they conjectured that treewidth alone is not enough, and that the problem is W[1]|-hard
with respect to this parameterization. In this thesis, we resolve this conjecture. Gaikwad
and Maity [61] gave a polynomial kernel for 7,,1-FREE EDGE DELETION parameterized by
k + h where k is the solution size. As T,41-FREE EDGE DELETION is NP-hard for every
h > 3 |44], it is not possible to obtain an FPT-algorithm parameterized by h alone (unless
P=NP). However, the parameterized complexity of this problem when parameterized by k

alone remains open.

MaxMin Separation Problems: Given the extensive study of the VERTEX COVER prob-
lem in parameterized complexity, the parameterized complexity MAXMIN VERTEX COVER
has naturally garnered significant attention [129] 19} [1301[3]. More recently, MAXMIN FEED-
BACK VERTEX SET problem has also been explored in [41] O8], where several faster FPT
algorithms are proposed. The MAXMIN DOMINATING SET problem has been studied in
[2] 4] 191 42], and its edge variant, the MAXMIN EDGE DOMINATING SET, is addressed
in [108]. Additionally, MAXMIN and MINMAX formulations have been investigated for a
range of other problems, including cut and separation problems [97} [40], knapsack problems
[78, 58], matching problems [24], and coloring problems [13]. We elaborate more on the

literature around MAXMIN versions of cut and separation problems in the later paragraph.

We remark that for most problems mentioned in the paragraph above, showing that they
are FPT parameterized by the solution size is not very difficult (though designing faster
FPT algorithms could be much challenging and require deep problem-specific combinatorial
insights). The reason is that it is easy to bound the treewidth of the input graph: find a
greedy packing of obstructions (edges for MAXMIN VERTEX COVER and cycles for MAXMIN
FEEDBACK VERTEX SET); if the packing size is at least k, then the instance is a yes-instance,
otherwise there exists a vertex cover (resp. feedback vertex set) of the input graph, of size at
most 2k (resp. O(klog k) from the Erdos-Posa theorem). In both case, the treewidth of the
graph is bounded by a function of k. Since these problems are expressible in Monadic Second

Order (MSO) Logic, from Courcelle’s theorem a linear-time FPT in k algorithm follows.

The key challenge in the study of cut and separation problems, in contrast to the

vertex/edge-deletion problems mentioned in the paragraph above, is that it is not always easy
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to bound the treewidth of the instances. Having said that the existing work on MAXMIN
versions of cut and separation problems are still based on treewidth win-win approaches as

explained below.

Hanaka et al. [81] studied the parameterized complexity of the MAXMIN SEPARATOR
problem. Here the input is only a connected graph G and a positive integer k, and the goal
is to find a minimal vertex set of size at least k whose deletion disconnects the graph. This
problem has an easy FPT algorithm parameterized by k£ based on a win-win approach: if the
input graph has large treewidth, then it has a large grid-minor which implies the existence
of a large solution; otherwise the treewidth is bounded and since the problem is expressible
in MSO, one can solve the problem in linear time on bounded treewidth graphs. Note that
this approach completely fails when we are looking for an st-separator (which is the problem
we attack in the present work) for fixed vertices s and ¢t. In particular, a large grid minor

does not necessarily imply a large st-separator.

In the same work Hanaka et al. designed an explicit FPT algorithm parameterized by
treewidth for the MAXMIN st-SEP problem and left open the question of determining the
parameterized complexity of MAXMIN st-SEP parameterized by the solution size k.

Later Duarte et al. [40] studied the edge-deletion variant of the MAXMIN st-SEP problem.
They termed it the LARGEST st-BOND problem and showed, amongst other results, that
it is FPT parameterized by the solution size k. At the core of this algorithm is another
treewidth-based win-win approach, which seems hard to generalize to the vertex-deletion

case.

The MAXIMUM MINIMAL st-SEPARATOR problem has been studied in the context of
parameterized complexity by Hanaka et al. [81]. Their work focuses on finding a mini-
mal st-separator with maximum weight in a vertex-weighted graph. They showed that the
MAXIMUM WEIGHT MINIMAL st-SEPARATOR problem is NP-hard, even on bipartite graphs
where all vertex weights are identical. The authors also explored structural parameteriza-
tion, providing an O*(tw®®™))-time deterministic algorithm based on tree decompositions,
where O* notation omits polynomial factors of n. Additionally, they improved the algo-
rithm using a rank-based approach, achieving a running time of O(38.2¢)*. Finally, they
presented an O (9™ - W?)-time randomized algorithm to determine whether there exists a

minimal st-separator, where W is its weight and tw is the treewidth of the graph G.
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Duarte et al. [40] examined the LARGEST st-BOND problem, where given an undirected
graph G, two vertices s,t € V(G), and a positive integer k, the task is to find a subset S C V'
such that s € S, t ¢ S, both G[S] and G[V \ S| are connected, and |0(S)| > k, where 9(5)
represents the set of edges with one endpoint in S and the other in V' '\ S. They proved
that the LARGEST st-BOND is NP-complete even on planar bipartite graphs, and showed
that it does not admit a constant-factor approximation algorithm unless P = NP. The paper
also provided an FPT algorithm parameterized by the solution size. The paper presented
several FPT algorithms parameterized by different structural parameters such as treewidth,

cliquewidth, and twin cover as well.

1.8 An Overview of the Thesis

In Chapter 2, we provide a brief introduction to Parameterized Complexity. We formally
define parameterized problems, fixed-parameter tractable algorithms, and kernelization. Ad-
ditionally, we introduce the concept of fixed-parameter intractability and the W-hierarchy.

Finally, we list all the structural graph parameters used in the thesis.

In Chapter 3, we investigate the computational complexity of the HARMLESS SET prob-
lem. This chapter is based on the papers |68 [65]. We show that the problem is W[1]-hard
when parameterized by several restrictive structural parameters, including the feedback ver-
tex set number, pathwidth, treedepth, and cluster vertex deletion number of the input graph.
On the positive side, we present fixed-parameter tractable (FPT) algorithms when the prob-
lem is parameterized by vertex integrity, neighborhood diversity, or twin cover. Additionally,
we establish an upper bound on the complexity by providing an XP algorithm when param-

eterized by clique-width.

In Chapter 4, we analyze the computational complexity of the DEFENSIVE ALLIANCE
problem. This chapter is based on the paper [61]. We prove that the problem is W[1]|-hard
when parameterized by several restrictive structural parameters, including the feedback ver-
tex set number, pathwidth, treewidth, treedepth, and clique-width of the input graph, even
under the restriction to bipartite graphs. Furthermore, we show that when parameterized
by the vertex cover number, the problem does not admit a polynomial compression unless
coNP C NP /poly. We also establish that it cannot be solved in 2°™ time unless the Expo-
nential Time Hypothesis (ETH) fails, and that the problem remains NP-complete on circle

22



graphs. Notably, the constructions used in our hardness proofs extend to problem variants

that require finding defensive alliances of exactly a given size.

In Chapter 5, we focus on the LOCALLY MINIMAL DEFENSIVE ALLIANCE problem. This
chapter is based on the paper [69]. We demonstrate that the problem is fixed-parameter
tractable (FPT) when parameterized by the solution size. For Cjs-free and Cy-free graphs
with a minimum degree of at least 2, we provide a kernel of size k°*). Additionally, when
restricted to planar graphs with a minimum degree of at least 2, we design an algorithm

with a running time of EOWVR oM, establishing efficient approaches for these graph classes.

In Chapter 6, we present the main contributions related to the GLOBALLY MINIMAL
DEFENSIVE ALLIANCE problem. This chapter is based on the papers [63] [64]. We show
that the problem is fixed-parameter tractable (FPT) when parameterized by neighborhood
diversity. On the other hand, we establish that the problem does not admit a polynomial
kernel when parameterized by the vertex cover number. Furthermore, we prove that the
problem is W[1]-hard when parameterized by several restrictive structural parameters, in-
cluding the feedback vertex set number, pathwidth, treewidth, and treedepth of the input
graph. Additionally, we demonstrate that deciding whether a given vertex v € V(G) is part

of a globally minimal defensive alliance in G is NP-complete.

In Chapter 7, we analyze the computational complexity of the OFFENSIVE ALLIANCE
problem. This chapter is based on the papers [60] [67]. We prove that the problem is
NP-complete, even when restricted to bipartite, chordal, split, and circle graphs. Further-
more, we establish that the problem is W[1]-hard when parameterized by several restrictive
structural parameters, including the feedback vertex set number, treewidth, pathwidth, and
treedepth of the input graph, implying that it is not fixed-parameter tractable (FPT) unless
FPT = W][1]. This resolves an open question posed by Bernhard Bliem and Stefan Woltran
(2018) regarding the complexity of OFFENSIVE ALLIANCE parameterized by treewidth. This
result is particularly noteworthy because most “subset problems” that are FPT when param-
eterized by solution size are also FPT for treewidth [33], and OFFENSIVE ALLIANCE is
computationally easy on trees. On the positive side, we show that the problem can be solved
in O*(ve(G)P0e@) time, where ve(G) is the vertex cover number of the input graph, and
it admits an FPT algorithm when parameterized by the vertex integrity of the input graph.
Additionally, we establish a lower bound based on the Exponential Time Hypothesis (ETH),
proving that the OFFENSIVE ALLIANCE problem cannot be solved in 2°(™ time, even when
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restricted to bipartite graphs, unless ETH fails.

In Chapter 8, we investigate the F~-FREE EDGE DELETION problem, a generalized frame-
work for edge-deletion tasks in graphs. This chapter is based on the papers |65 62]. The
problem asks whether it is possible to remove at most k edges from an input graph G such
that the resulting graph avoids all forbidden subgraphs from a given set F. Our results ex-
plore both the algorithmic and complexity aspects of this problem. Specifically, we show that
the problem is W|1]-hard when parameterized by the treewidth of the input graph combined
with the size of F. Additionally, we establish that it is W|2]-hard when parameterized by the
combined parameters of solution size, feedback vertex set number, and pathwidth. These
hardness results highlight the problem’s inherent complexity across a range of structural

parameters, advancing the theoretical understanding of edge-deletion problems.

In Chapter 9, we focus on the T}, ,;-FREE EDGE DELETION problem, a special case of
F-Free Edge Deletion, where F is the set of all trees with A + 1 vertices. This chapter is
based on the paper [66]. This problem is motivated by practical applications in epidemiology,
where minimizing the spread of diseases can be modeled by controlling connected component
sizes in networks. We prove that the problem is NP-complete even when restricted to planar
graphs. Further, we show that the problem is W[1]-hard when parameterized by treewidth
alone, thereby resolving an open conjecture by Enright and Meeks (2018). Additionally, we
study its directed variant and prove that the problem is W|2|-hard when parameterized by
solution size on directed acyclic graphs. These results underscore the complexity of this

edge-deletion task and provide key insights into its parameterized complexity.

In Chapter 10, we investigate the parameterized complexity of the MAXIMUM MINIMAL
st-SEPARATOR and MAXIMUM MINIMAL ODD CYCLE TRANSVERSAL (OCT) problems.
This chapter is based on the paper [59]. We establish that both problems are fixed-parameter
tractable (FPT) when parameterized by the solution size.

In Chapter 11, we present our conclusions and discuss several open problems.
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Chapter 2

Preliminaries

In this chapter, we present some basic definitions in graph theory and introduce terminologies

that are used in this thesis. We also cover the basics of parameterized complexity.

2.1 Graph Theory

In this section, we provide some basic definitions from graph theory. For standard notations
and definitions in graph theory, we refer to West [127]. Let G = (V, E') denote a finite, simple,
and undirected graph. We denote by V(G) and E(G) its vertex and edge set, respectively.
For a vertex v € V, we use N(v) = {u : (u,v) € E(G)} to denote the open neighbourhood of
vin G, and Nv] = N(v)U{v} to denote the closed neighbourhood of v. The degree d(v) of a
vertex v € V(G) is [N (v)]. For asubset S C V(G), weuse Ng(v) ={u €S : (u,v) € E(G)}
to denote the (open) neighbourhood of vertex v in S, and dg(v) to denote the number of
neighbours of vertex v in S. We denote by S¢ the set of vertices in G — S. For s,t € V(G),
an st-path in G refers to a path between s to ¢ in G. For subsets S, T C V(G), an ST-path is
a path between a vertex of S to a vertex of T'. A graph H is said to be a subgraph of a graph
Gif V(H) CV(G) and E(H) C E(G), denoted by H C G. Given a graph G = (V. E) and a
subset of vertices U C V, the induced subgraph G[U] is the subgraph of G that includes all
vertices in U and all edges in I that have both endpoints in U. We also define d-scattered

set in graphs which will be used later.
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Definition 2.1.1 (d-scattered set). Let G = (V, E) be a graph and let d be a positive integer.
A set S C V is called a d-scattered set if for every pair of distinct vertices u,v € S, the

distance between u and v in G is greater than d, i.e., distg(u,v) > d.

2.1.1 Graph Types

A directed graph (or digraph) is a graph where the edges have a direction.

A graph is said to be connected if there is a path between every pair of vertices. If
the graph is not connected, it is called a disconnected graph. The maximal connected

subgraphs are called connected components of a graph.

A graph is called a tree if it is connected and acyclic. The height of a rooted tree is

the maximum number of vertices on a path from root to a leaf.

A graph is called bipartite if its vertices can be partitioned into two sets V4 and V5 such

that every edge has one endpoint in V; and the other in V5.

A graph G is said to be a chordal graph if every cycle of G of length at least four has

a chord.

A split graph is a special kind of chordal graph where its vertex set V(G) can be
partitioned into two subsets V; and V; such that the graph G[V4] is a clique and G[V53]

is an independent set.

A circle graph is the intersection graph of a set of chords of a circle. That is, it is an
undirected graph whose vertices can be associated with chords of a circle such that two

vertices are adjacent if and only if the corresponding chords cross each other.

A planar graph is a graph that can be embedded in the plane, that is, it can be drawn
on the plane in such a way that its edges intersect only at their endpoints. In other

words, it can be drawn in such a way that no edges cross each other.

An apex graph is a graph that can be made planar by the removal of a single vertex.

The deleted vertex is called an apex of the graph.
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2.1.2 Brekable and Unbreakable Graphs

A pair (X,Y), where X UY = V(G), is called a separation if E(X \Y,Y \ X) = 0. The
order of (X,Y) is [ X NY|. If there exists a separation (X,Y’) of order at most & such that
|IX\Y|>gqgand |Y \ X| > ¢, then G is (q, k)-breakable and the separation (X,Y") is called
a witnessing separation for the (g, k)-breakability of G. Otherwise, G is (q, k)-unbreakable.

2.1.3 Monadic Second Order Logic

The syntax of Monadic Second Order Logic (MSO) formula for graphs includes the logical
connectives V,A\,—, => , <= , variables for vertices, edges, sets of vertices, and sets of

edges, the quantifiers V and 4, and five binary relations:

1. uw € U, where u is a vertex variable and U is a vertex set variable;
2. d € D, where d is an edge variable and D is an edge set variable;

3. inc(d, u), where d is an edge variable, u is a vertex variable, and the interpretation is
that the edge d is incident to u;

4. adj(u,v), where u and v are vertex variables, and the interpretation is that v and v

are adjacent;

5. equality of variables representing vertices, edges, vertex sets, and edge sets.

Counting Monadic Second Order Logic (CMSO) extends MSO by including atomic sen-
tences testing whether the cardinality of a set is equal to ¢ modulo r, where ¢ and r are
integers such that 0 < ¢ < r and r > 2. That is, CMSO is MSO with the following atomic
sentence:

card,,(S) = true if and only if |S| =¢ mod r,

where S is a set. For our applications, we will restrict ourselves to MSO, whereas the next

proposition holds for the more general CMSO.
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2.2 Introduction to Parameterized Complexity

The time taken by an algorithm is a key measure of its efficiency. For many problems,
there are algorithms with time complexities that are polynomial in the size of the input.
However, for a large number of problems, no polynomial-time algorithm is known to exist.
The algorithms that are known for such problems are typically exponential in the size n
of the input. As a result, the time complexity grows much more rapidly with n, making
the problem computationally intractable even for relatively small instances. Downey and
Fellows [35] introduced the framework of Parameterized Complexity in early 90’s for dealing
with such hard problems. This framework measures the computational complexity of the
problem using the input size and an additional measurement which is called the parameter.

We refer to [31][36] for further details on parameterized complexity.

2.2.1 Fixed Parameter Tractability

A popular method to soften this exponential blow is to look at parameterized problems. Here,
every instance is attached with a parameter k, thereby expanding the set of all instances to

>* x N. Next, we move to the formal definition of parameterized problems.

Definition 2.2.1. [31] A parameterized problem I1 is a subset of ¥* x N, where ¥ is a (fized)
finite alphabet set. An instance of a parameterized problem is a tuple (x, k), where k is called

the parameter.

A parameterized problem now asks a Yes/No question, often linking z to k, and is charac-
terised by a subset L C »* x N of yes instances. Parameterized complexity introduces the
notion of fixed-parameter tractability (FPT), which classifies problems based on their com-
plexity with respect to the parameter. A problem is called FPT if there exists an algorithm
that solves it within a polynomial-time bound in the input size, multiplied by a function of
the parameter. This characterization allows for a fine-grained analysis of complexity. Here

we provide a formal definition of a fixed parameter tractable problem.

Definition 2.2.2. [31] A parameterized problem L C ¥* x N is called fized parameter
tractable if there exists an algorithm A , a computable function f : N — N, and a constant

¢ such that, given (x,k) € ¥* x N, the algorithm A correctly decides whether (x,k) € L in
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time bounded by f(k)-|(x,k)|¢. The complexity class containing all fixed parameter tractable
problems is called FPT.

2.3 Standard Techniques used to Design FPT Algorithms

There are several standard techniques for designing FPT algorithms. In this section, we

discuss a few of them that are primarily used in this thesis.

2.3.1 Kernelization

Kernelization stands as a cornerstone of parameterized complexity, offering a powerful tool
to process complex problem instances to their essential cores. Formally, a kernelization
algorithm takes as input an instance of a problem along with a parameter and outputs
an equivalent instance whose size is bounded by a polynomial function of the parameter.
This output, known as a kernel, retains the essence of the original problem while being
significantly smaller in size. Moreover, kernelization serves as a bridge between parameterized
complexity theory and practical algorithm design. By providing a systematic framework
for preprocessing problem instances, it allows the construction of fixed-parameter tractable
(FPT) algorithms that exhibit polynomial-time complexity when the parameter is considered

small. Now we provide a formal definition of Kernelization Algorithm.

Definition 2.3.1. [31] A kernelization algorithm, or simply a kernel, for a parameterized
problem @Q is an algorithm A that, given an instance (I, k) of Q, works in polynomial time
and return an equivalent instance (I', k") of Q. Moreover, we require that sizes(k) < g(k)

for some computable function g : N — N.

2.3.2 Bounded search trees

Branching search trees is also known as branching technique. Branching, is one of the sim-
plest and most commonly used techniques in parameterized complexity that uses in general
the idea of systematic search. Branching algorithms traverse a search tree representing

possible solutions to a given problem instance, with each node corresponding to a partial
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solution. At each branching point, decisions are made based on the current state of the
solution and the values of additional parameters, branching into subproblems that explore
different choices. This iterative process continues until either a solution is found or the
search space is exhausted, providing valuable information about the problem’s complexity.
Branching algorithms often exhibit exponential worst-case complexity, especially when ex-
ploring all possible branches of the search tree. This exponential growth arises from the
combinatorial explosion of choices at each branching point, making exhaustive exploration
impractical for large instances. The complexity of branching algorithms depends upon both
the branching factor and the depth of the search tree. A high branching factor or depth can
significantly increase the number of nodes explored, leading to longer execution times and

increased memory requirements.

2.3.3 Parameterized Reduction

Lets recall that the idea of polynomial time reduction in classical complexity allows us to
prove NP-hardness results. In the polynomial time reduction algorithm, we map an instance
of problem A to an equivalent instance of problem B in polynomial time. If there is a poly-
nomial time reduction from problem A to a problem B then it implies that if a problem B
can be solved in polynomial time then the problem A also admits a polynomial time algo-
rithm. The underlying idea in the parameterized reduction is similar, except here we have
to maintain the size of a parameter in the reduced problem. Formally, a parameterized re-
duction maps instances of one parameterized problem to instances of another, such that the
transformed instance preserves the parameterized complexity of the original. This preserva-
tion ensures that the inherent difficulty captured by additional parameters remains intact,
which helps us to analyze the complexity landscape of problems through a parameterized

lens. Here, we provide a formal definition of parameterized reduction.

Definition 2.3.2. [31] Let A, B C ¥* x N be two parameterized problems. A parameterized
reduction from problem A to B is an algorithm that, given an instance (z,k) of A, outputs
an instance (', k") of B such that

1. (z,k) is a yes instance of A if and only if (2, k') is a yes instance of B,

2. k' < g(k) for some computable function g, and
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3. the running time is f(k).|2|°0) for some computable function f.

The parameterized reduction provides a way to prove that a problem is FPT in the following

way.

Theorem 2.3.1. 31 If there is a parameterized reduction from problem A to B and B is
FPT, then A is FPT as well.

2.3.4 The ILP Technique

This technique is based on a computational problem called integer linear programming (ILP).
A special and important case of the parameterized reduction technique is to map a problem
to an integer linear programming (ILP) problem. Integer linear programming (ILP) is de-

fined as follows:

p-ILP
Input: A matrix A € Z™*P, and vectors b € ZP and ¢ € ZP.

Question: Find a vector x € ZP that satisfies the m inequalities, that is, A -z > b.

Parameter: p, the number of variables.

Lenstra [99] showed that p-ILP is FPT with running time doubly exponential in p, where
p is the number of variables. Later, Kannan [87] proved an algorithm for p-ILP running in
time p©® . In our algorithms, we need the optimization version of p-ILP rather than the
feasibility version. We state the minimization version of p-ILP as presented by Fellows et

al. [47]. The problem and the result are formally stated as follows.

p-OpT-1LP
Input: A matrix A € Z™*P, and vectors b € ZP and c € ZP.
Question: Find a vector x € ZP that minimizes the objective function ¢’ -z and satisfies

the m inequalities, that is, A-x > b.

Parameter: p, the number of variables.

Lemma 2.3.2 (Fellows et al. [47]). p-OPT-ILP can be solved using O(p*°P+°®). L.log(M N))

arithmetic operations and space polynomial in L. Here L is the number of bits in the input,
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N s the mazimum absolute value any variable can take, and M is an upper bound on the

absolute value of the minimum taken by the objective function.

Given any problem P with parameter k£, we must try to reformulate it as an ILP problem
with at most f(k) variables for some computable function f in order to apply the ILP
technique. This is the most crucial requirement for this technique. If then, substituting
L, M and N gives a running time polynomial in n, we shall obtain an FPT algorithm for
the problem P.

2.4 Standard Techniques used to Design Lower Bounds

In this section, we will focus on the tools used in this thesis to establish lower bounds on the

complexity of certain computational problems.

2.4.1 The W-hierarchy

As discussed in the previous section, the first technique is parameterized reduction. We
know that given a parameterized reduction from problem A to B implies that if the problem
B is FPT then so is problem A. Similar to the theory of NP-completeness, we can also say
that problem A is at least as hard as problem B. Note that, if we assume that the problem
A is not FPT then it implies that the problem B is not FPT as well. If we accept as a
working hypothesis that CLIQUE is not fixed-parameter tractable, then given a reduction
from CLIQUE to other problems suggests that the other problem is not fixed-parameter
tractable either. Lets look at some simple examples. There is a simple parameterized
reduction from CLIQUE to INDEPENDENT SET. This implies that CLIQUE is at least as hard
as INDEPENDENT SET. Let us recall that given any two NP-complete problems, there is
always exists a polynomial time reduction from one to the other. This kind of phenomenon
seem to fail in the realm of parameterized complexity. We know that there is parameterized
reduction from CLIQUE to DOMINATING SET but there is no known parameterized reduction
from DOMINATING SET to CLIQUE. This suggests that there are different levels in the
parameterized complexity of hard problems. More details of W-hierarchy can be in Chapter

13 in [31]. As far as this thesis is concerned, we are mainly interested in whether given
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problem and a parameter, does there exists a FPT algorithm or not. If there is no FPT
algorithm then we do not pay much attention to the different levels of hardness in the W-
hierarchy. The following theorem are used to provide a number of lower bounds on the

complexity of some problems.
Theorem 2.4.1. [35] INDEPENDENT SET is W/1/-hard.

Theorem 2.4.2. [31]/ DOMINATING SET, SET COVER, and HITTING SET are W/[2[-hard.

It is important to note that the above theorem are true under the assumption that FPT#W]|1]
and not P#£NP.

2.4.2 Lower Bounds based on Exponential Time Hypothesis

The Exponential Time Hypothesis (ETH) is a conjecture stating that n-variable 3-SAT can-
not be solved in time 2°™. Standard techniques for designing FPT algorithms—such as
branching, kernelization, color coding, and iterative compression—typically yield algorithms
klogk) . nOM) o1 even 2PN () . nOM) " These

forms suggest that FPT problems admit an internal hierarchy based on the parameter de-

with running times of the form 20%) . p©) 20( n
pendence of their running times. The ETH conjecture serves as a useful tool in establishing
this hierarchy. It allows us to show that, for many problems, the known FPT algorithms
are essentially optimal under ETH. In the context of parameterized complexity, ETH is fre-
quently used to prove lower bounds of the form that a problem cannot be solved in time

20(k) . nO) " golk) . nOW) "or even f(k) - n°*) for any computable function f.

2.4.3 Lower bounds on Kernelization

As mentioned in the previous section, kernelization is one of the important tool to construct
FPT algorithms. There are number of classical problems for which polynomial kernels have
been constructed. For example, VERTEX COVER admits a kernel of size 2k, and FEEDBACK
VERTEX SET admits a kernel of size O(k?). The list is quite long. One might ask whether,
given a parameterized problem, it is always possible to construct a kernel of polynomial
size. Unfortunately, the answer is no. There are many ways to prove the non-existence

of a polynomial kernel. Here, we discuss one such method called Polynomial Parameter

33



Transformation(PPT) which is used multiple times in this thesis. This technique allows us
to transfer the hardness from one problem to another. To do this, we provide the following

definition.

Definition 2.4.1 ([53]). Let P,Q C X* x N be two parameterized problems. An algorithm A
is called a polynomial parameter transformation from P to Q if, given an instance (x,k) of
problem P, A works in polynomial time and outputs an equivalent instance (z', k") of problem
Q, that is, (z,k) € P if and only if (2’ k') € Q, such that k' < p(k) for some polynomial

p(.)-

The above definition allows us to state the following.

Theorem 2.4.3 (|53]). Let P,Q C X* x N be two parameterized problems and assume there
exists a polynomial parameter transformation from P to Q). Then, if P does not admit a

polynomial compression, neither does Q).

We refer to [53] for further details on kernelization.

2.5 Structural Graph Parameters

In this section, we recall the definitions of the structural graph parameters discussed in this

thesis and provide information on how these parameters are related to one another.

Definition 2.5.1. A set S C V(G) is a vertex cover of G = (V, E) if each edge in F has at
least one endpoint in S. The size of a smallest vertex cover of G is the vertex cover number
of G.

Next, we define Twin Cover, Neighbourhood Diversity, and Vertex Integrity of a graph.

According to Ganian [73], “The twin-cover is a direct generalization of vertex cover towards
richer graph classes, including dense graph classes (which have unbounded treewidth)." Here

we relax the definition of vertex cover so that not all edges need to be covered.
Definition 2.5.2. [73] An edge (u,v) € E(G) is a twin edge of G if N[u] = N[v].
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Definition 2.5.3. [73] A set X C V(G) is a twin-cover of G if every edge in G is either
twin edge or incident to a vertex in X. We then say that G has twin-cover number k if k is

the minimum possible size of a twin-cover of G.

U3 Ve
@) Q
V2
U1
O O
Us U7

(a) (b)

Ug

Figure 2.1: (a) A minimum vertex cover (size 5—depicted in black) (b) A minimum twin-cover
(size 2—depicted in blue). The twin edges are shown in red.

Two different vertices u, v are called true twins if Nju| = N[v]. Likewise, u, v are called
false twins if N(u) = N(v). In general, u, v are called twins if they are either true twins or

false twins. If they are twins, we say that they have the same neighbourhood type.

Definition 2.5.4. [96] A graph G = (V, E) has neighbourhood diversity at most d, if there
exists a partition of V into at most d sets (we call these sets type classes) such that all the

vertices in each set have the same neighbourhood type.

If the neighbourhood diversity of a graph is bounded by an integer d, then there exists a
partition {C7,Cs,...,Cy} of V(G) into d type classes. We would like to point out that
it is possible to compute the neighbourhood diversity of a graph in linear time using fast
modular decomposition algorithms [124]. Notice that each type class could either be a clique
or an independent set by definition. For algorithmic purposes it is often useful to consider
the type graph H of G, where each vertex of H is a type class in GG, and two vertices C;
and C; are adjacent if and only if every vertex of C; is adjacent to every vertex of Cj
in G. It is not difficult to see that there will be either no edges between C; and Cj or
every vertex of C; is adjacent to every vertex of Cj. The key property of graphs of bounded
neighbourhood diversity is that their type graphs have bounded size. For example, a graph
GG with neighbourhood diversity four and its corresponding type graph H are illustrated in

Figure

35



G H

Figure 2.2: A graph G with neighbourhood diversity 4 and its corresponding type graph H.

Next, we move to vertex integrity. This vulnerability measure was introduced by Barefoot et
al. [7] in 1987. For an overview of structural results on vertex integrity, we refer the reader

to a survey on vertex integrity by Bagga et al. [6].

Definition 2.5.5. |7 The vertex integrity of a graph G, denoted vi(G), is the minimum
integer k satisfying that there is X C V(G) such that | X|+|V(C)| < k for each component
C of G—X. We call such S a k-vi-set of G.

Gima et al. [77] described an equivalence relation among components. For a vertex set
X of G, we define an equivalence relation ~¢ x among components of G — X by setting
Cy ~¢.x Cy if and only if there is an isomorphism ¢ from G[X UV (C})] to G[.X U V(Cy)]
that fixes X; that is, g|x is the identity function. When C ~¢ x Cs, we say that C; and Cy
have the same (G, X)-type (or just the same type if G and X are clear from the context).

See Figure
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Figure 2.3: The components Cy and C3 of G — X have the same (G, X)—type.

This equivalence relation induces a set of equivalence classes Cq,Cs,.... We can choose a

representative of each equivalence class.

Next, we define cluster vertex deletion number of a graph. One can observe that this is a

generalization of twin cover in dense graphs.

Definition 2.5.6. [34)] The cluster vertex deletion number of a graph is the minimum num-

ber of its vertices whose deletion results in a disjoint union of complete graphs.

JAYA

Figure 2.4: The red vertex form a cluster vertex deletion set of the given graph.

An example of a cluster vertex deletion set is given in Figure[2.4]

Next, we will define feedback vertex set and treedepth of a graph.

Definition 2.5.7. A feedback vertex set of a graph G is a set of vertices whose removal turns
G into a forest. The minimum size of a feedback vertex set in G is the feedback vertex set
number of G, denoted by fvs(G).

Figure 2.5: The red vertices form a feedaback vertex set of the given graph.

An example of a feedback vertex set is given in Figure
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A rooted forest is a disjoint union of rooted trees. Given a rooted forest F, its transitive
closure is a graph H in which V (H) contains all the nodes of the rooted forest, and E(H) con-
tain an edge between two vertices whenever those two vertices form an ancestor-descendant

pair in the forest F.

Definition 2.5.8. The treedepth of a graph G is the minimum height of a rooted forest F
whose transitive closure contains the graph G as a subgraph. It is denoted by td(G).

Example is given in figure

Figure 2.6: The input graph is on the left and a corresponding rooted forest is on the right.

Next, we review the concept of a tree decomposition introduced by Robertson and Seymour

[116]. Treewidth is a measure of how “tree-like” the graph is.

Definition 2.5.9 (Robertson and Seymour [116]). A tree decomposition of a graph G =
(V,E) is a tree T' together with a collection of subsets X, (called bags) of V' labeled by the
nodes t of T' such that J,. Xy =V and (1) and (2) below hold:

1. For every edge (u,v) € E(G), there is some t such that {u,v} C X;.
2. (Interpolation Property) If t is a node on the unique path in T from t; to ts, then
Xy, N Xy, € X,

Definition 2.5.10 (Robertson and Seymour [116]). The width of a tree decomposition is the
mazximum value of | X;| — 1 taken over all the nodes t of the tree T of the decomposition. The

treewidth tw(G) of a graph G is the minimum width among all possible tree decompositions
of G.
Example 1. Figure gies an example of a tree decomposition of width 2.

Definition 2.5.11. If the tree T of a tree decomposition is a path, then we say that the tree
decomposition is a path decomposition. The pathwidth pw(G) of a graph G is the minimum

width among all possible path decompositions of G.
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Figure 2.7: Example of a tree decomposition of width 2

Finally, we define cliquewidth of a graph.

Definition 2.5.12. The clique-width of a graph G, denoted by cw(G), is the minimum

number of labels needed to construct G using the following four operations:

1. Create a new graph with a single vertex v with label i (written i(v)).
2. Take the disjoint union of two labelled graphs G1 and G (written G & G3).

3. Add an edge between every vertex with label i and every vertex with label j, i # j

(written n; ;).

4. Relabel every vertex with label i to have label j (written p;_;).

We say that a construction of a graph G with the four operations is a c-expression if it uses at
most ¢ labels. Thus the clique-width of G is the minimum c for which G has a c-expression.

A c-expression is a rooted binary tree T such that

1. each leaf has label i for some i € {1,...,c},
2. each non-leaf node with two children has label ®, and
3. each non-leaf node with only one child has label p;_; orn;; (1,7 € {1,...,c}, i # j).
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Example 2. Consider the graph P,, which is simply a path on n vertices. Note that
cw(Py) =1 and cw(P,) = cw(P;) = 2. Now consider a path on four vertices vy, v, v3, vy, in
that order. Then this path can be constructed using the four operations (using only three

labels) as follows:

M3,2(3(va) ® p3sa(pa—s1(M32(3(v3) ® N21(2(v2) © 1(v1))))))-

Figure|2.8|shows a 3-expression of P,. This construction can readily be generalized to longer

N3,2

Figure 2.8: A 3-expression of Pj.

paths for n > 5. It’s easy to see that cw(P,) < 3 for all n > 4.

A c-expression represents the graph represented by its root. A c-expression of an n-vertex
graph G has O(n) vertices. A c-expression of a graph is irredundant if for each edge {u, v},
there is exactly one node n;; that adds the edge between u and v. It is known that a c-
expression of a graph can be transformed into an irredundant one with O(n) nodes in linear

time [30]. From here on, we assume we are given an irredundant c-expression.

Computing the clique-width and a corresponding c-expression of a graph is NP-hard. For
¢ < 3, we can compute a c-expression of a graph of clique-width at most ¢ in O(n?m) time,
where n and m are the number of vertices and edges, respectively. For fixed ¢ > 4, it is
not known whether one can compute the clique-width and a corresponding c-expression of
a graph in polynomial time. On the other hand, it is known that for any fixed ¢, one can
compute a (27! —1)-expression of a graph of clique-width ¢ in O(n?) time. For more details,

see |86].
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Figure shows how these parameters are related to each other.

pw fvs

Figure 2.9: Relationship between vertex cover [vc| (see Definition , neighbourhood
diversity [nd] (see Definition , twin cover [tc| (see Definition , modular width
[mw]| (see [107]), cluster vertex deletion number [cvd] (see Definition [2.5.6), feedback vertex
set [fvs| (see Definition , pathwidth [pw] (see Definition , treewidth [tw] (see
Definition and clique width [cw]| (see Definition [2.5.12)). Note that A — B means
that there exists a function f such that for all graphs, f(A(G)) > B(G).
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Chapter 3

Harmless Set

In this chapter, we study the HARMLESS SET problem from a parameterized complexity
perspective. Given a graph G = (V, E), a threshold function ¢ : V — N and an integer k,
we study HARMLESS SET, where the goal is to find a subset of vertices S C V of size
at least k such that every vertex v € V has fewer than t(v) neighbours in S. On the
positive side, we obtain fixed-parameter algorithms for the problem when parameterized by
the neighbourhood diversity, the twin-cover number and the vertex integrity of the input
graph. We complement two of these results from the negative side. On dense graphs, we
show that the problem is W[1]-hard parameterized by cluster vertex deletion number — a
natural generalization of the twin-cover number. We show that the problem is W][1]-hard
parameterized by a wide range of fairly restrictive structural parameters such as the feedback
vertex set number, pathwidth, and treedepth — a natural generalization of the vertex integrity.
We thereby resolve one open question stated by Bazgan and Chopin (Discrete Optimization,
2014) concerning the complexity of HARMLESS SET parameterized by the treewidth of the
input graph. We also show that HARMLESS SET for a special case where each vertex has
the threshold set to half of its degree (the so-called MAJORITY HARMLESS SET problem) is
W][1]-hard when parameterized by the treewidth of the input graph. Given a graph G and
an irredundant c-expression of GG, we prove that HARMLESS SET can be solved in XP-time
when parameterized by clique-width. This chapter is based on the papers [65] [68].
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3.1 FPT via Integer Linear Programming

In this section, we study the parameterized complexity of HARMLESS SET with respect to
various structural parameters. Drange, Muzi and Reidl [38] proved that HARMLESS SET
is fixed-parameter tractable when parameterized by the vertex cover number of the input
graph. We present FPT algorithms for HARMLESS SET parameterized by the neighbour-
hood diversity, twin-cover number, and vertex integrity of the input graph. Studying the
parameterized complexity of HARMLESS SET with respect to these parameters improves the
understanding of the boundary between tractable and intractable parameterizations. Our
algorithms use an integer linear programming (ILP) subroutine. ILP is a well known frame-
work for formulating problems and a powerful tool for the development of fixed-parameter

algorithms for optimization problems.

Suppose A and B are two parameterized problems. It is known that if A fpt-reduces to
B and B is fixed-parameter tractable, then A is fixed-parameter tractable as well. In this
section, we shall let B be the classical Integer Linear Programming (ILP) problem, and show
how FPT algorithms can be designed for HARMLESS SET, via the ILP method. Given any
problem P with parameter k, we must try to reformulate it as an ILP problem with at most
f (k) variables for some computable function f in order to apply the ILP technique. This is

the most crucial requirement for this technique.

3.1.1 HARMLESS SET Parameterized by Neighbourhood Diversity

Let us first recall the definition of neighbourhood diversity. Two different vertices u, v are
called true twins if N[u] = N[v]. Likewise, u, v are called false twins if N(u) = N(v). In
general, u, v are called twins if they are either true twins or false twins. If they are twins,

we say that they have the same neighbourhood type.

Definition 3.1.1 (Lampis [96]). has neighbourhood diversity at most d, if there exists a
partition of V' into at most d sets (we call these sets type classes) such that all the vertices

in each set have the same neighbourhood type.

The following result explains why the vertices with low thresholds are included in the solution.
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Lemma 3.1.1. Let C; be a type class and (v, v, ..., v,,) be an increasing ordering of V()
according to threshold values, that is, t(v;) < t(vg) < -+ < t(v,,). Let S be a maximum size
harmless set in G and S; = C;NS. Then S" = (S\ S;) U{v1,vs,...,v5,} is also a maximum

size harmless set in G.

Proof. Clearly, |S| = |S’|. To show S’ is a harmless set, it is enough to show that each vertex
v in C; has fewer than ¢(v) neighbours in S’. Recall that C; is either an independent or a
clique type class and every vertex in C; has the same neighbourhood in G. We consider two

cases:

Case 1: Suppose that v € {v1,...,v}s,}, that is, v has a smaller threshold value. If v € S,
then it will remain in S’. Therefore dg/(v) = ds(v) < t(v). If v ¢ S, then to obtain S’ from
S we remove a vertex v’ € S\ {vi,...,vs,} having a larger threshold value t(v') > t(v)
from S and include v in S. Note that dg/(v) = dg(v) < t(v) if C; is an independent type
class whereas dg/(v) = dg(v) — 1 < t(v) if C; is a clique type class. Therefore v satisfies the
threshold condition.

Figure 3.1: Illustration of Case 1 where v is a vertex with small threshold value but outside .S.
Here C; is a clique type class and S; = SN C; is shown in yellow. Clearly dg (v) = dg(v) — 1.
If v satisfies the threshold condition for S then it also satisfies the threshold condition for
S’.

Case 2: Suppose that v € {v|g,41,. ..,V }, that is, v has a larger threshold value. If v € S
then v ¢ ', therefore dg/(v) = dg(v) < t(v). If v € S then, to obtain S’ from S we remove
v from S and include a vertex v € {vi,vs,...,v;5,}\S having a smaller threshold value
t(v') < t(v). If C; is an independent type class then dg (v) = dg(v) < t(v). If C; is a clique
type class, then we have dg(v') = dg(v) + 1 and also dg/(v) = ds(v) + 1. It implies that
ds/(v) = dg(v) + 1 =dg(v') < t(v') < t(v). That is, v has fewer than ¢(v) neighbours in 5.

Therefore, S’ is a harmless set. O
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Figure 3.2: Illustration of Case 2 where v is a vertex with a larger threshold value but inside
S. Here C; is a clique type class and S; = S N C; is shown in yellow.

In this section, we prove the following theorem:

Theorem 3.1.2. HARMLESS SET can be solved in time d°@ - n®M) where d is the neigh-
bourhood diversity of the input graph.

Given a graph G = (V, F) with neighbourhood diversity nd(G) < d, we first find a parti-
tion of the vertices into at most d type classes C1, ..., Cy. Let C be the set of all clique type
classes and Z be the set of all independent type classes. The case where some C; are single-
tons can be considered as cliques or independent sets. For simplicity, we consider singleton

type classes as independent sets.

ILP formulation: Our goal here is to find a largest harmless set S of GG. For each C;, we
associate a variable x; that indicates |S N C;| = x;. As the vertices in C; have the same
neighbourhood, the variables z; determine S uniquely, up to isomorphism. The threshold
t(C;) of a type class C; is defined to be

t(C;) = min{t(v) | v € C;}.

Let «(C;) be the number of vertices in C; with threshold value ¢(C;). We define C. = {C; €
C|lxz < a(C)}and C> = {C; € C | & > «(C;)}. We next guess if a clique type class
C; belongs to C. or C>. There are at most 2¢ guesses as each clique type class C; has two
options: either it is in C. or in C>. We reduce the problem of finding a maximum harmless
set to at most 2¢ integer linear programming problems with d variables. Since integer linear
programming is fixed-parameter tractable when parameterized by the number of variables
[99], we conclude that our problem is FPT when parameterized by the neighbourhood di-
versity d. We consider the following cases based on whether C; is in Z,C. or C>. See Figure
for an illustration.
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Case 1: Assume that C; is in 7.

Lemma 3.1.3. Let C; be an independent type class. Let ug be a vertex in C; with thresh-
old ¢(C;). Then every vertex u in C; has fewer than t(u) neighbours in S if and only if v,
has fewer than ¢(C;) neighbours in S.

Proof. Suppose each u € C; has fewer than ¢(u) neighbours in S. Then obviously uy € C;
has fewer than t(ug) = t(C;) neighbours in S. Conversely, suppose ug has fewer than t(C;)
neighbours in S. Let u be an arbitrary vertex of C;. As uw and ug are two vertices in the

same type class C;, we have dg(u) = dg(ug). Moreover, for each u € C;, we have t(C;) < t(u)

by definition of ¢(C;). Therefore, dg(u) = ds(up) < t(C;) < t(u). O
Note that dg(ug) = >, ;. By Lemma|3.1.3| every vertex u in C; has fewer than ¢(u)
C;eNE(Cy)

neighbours in S if and only if

C;eNg(Cy)

() (i) (i41)

Figure 3.3: (i) Case 1: An independent type class C; with ¢(C;) = 8 and z; = 3. (i7) Case
2: A clique type class C; in C. with ¢(C;) = 8, a(C;) = 3 and x; = 2 < a(C;). (iii) Case 3:
A clique type class C; in C> with ¢(C;) =8, a(C;) = 3 and z; = 4 > «(C;); SN C; is shown
in yellow.

Case 2: Assume that C; is in C.. That is, C; is a clique type class and x; < a(C;).
Assumption z; < a(C;) ensures that there exists at least one vertex in SN C; with threshold

HCy).

Lemma 3.1.4. Let C; € C. and ug be a vertex in S°N C; with threshold ¢(C;). Then every
vertex u in C; has fewer than #(u) neighbours in S if and only if 4 has fewer than ¢(C;)

neighbours in S.
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Proof. Suppose every vertex u in C; has fewer than ¢(u) neighbours in S. Then obviously
up has fewer than t(ug) = t(C;) neighbours in S. Conversely, suppose ug has fewer than
t(C;) neighbours in S. Let u be an arbitrary vertex of C;. If u € SN C;, then Lemma
and the condition x; < «(C;) ensure u has threshold ¢(C;). Note that dg(u) = dg(ug) — 1 <
t(Ci) — 1 < t(C;) = t(u). If u e S°NC;, then we have dg(u) = dg(ug) < t(C;) < t(u).

Therefore, every vertex in C; satisfies the threshold condition. n

Note that ds(ug) =x; + Y, ;. By Lemmal3.1.4] every vertex u in C; has fewer than
C;eNu(Cy)
t(u) neighbours in S if and only if

Case 3: Assume that C; is in Cs. That is, C; is a clique type class and x; > a(C;). By
Lemma|3.1.1] all the vertices with threshold ¢(C;) are inside S.

Lemma 3.1.5. Let C; € C> and ug be a vertex in S N C; with threshold #(C;). Then every
vertex u in C; has fewer than ¢(u) neighbours in S if and only if uy has fewer than ¢(C;)

neighbours in S.

Proof. Suppose every vertex u in C; has fewer than ¢(u) neighbours in S. Then obviously ug
has fewer than t(ug) = t(C;) neighbours in S. Conversely, suppose ug has fewer than ¢(C;)
neighbours in S. Let u be an arbitrary vertex of C;. If u € SN C;, then dg(u) = dg(ug) <
t(C;) < t(u). Suppose u € S°N C;. Note that such an element u may not always exist,
it is possible that all vertices in C; are included in S (that is, z; = |C;]). Let us assume
that such u exists. Since w is outside the solution and by Lemma all the vertices
with threshold ¢(C;) are inside the solution, we get t(u) > t(C;) + 1. It is easy to note that
ds(u) = dgs(ug) +1 < t(C;) + 1 < t(u). O

Here dg(ug) = (z; —1)+ >, ;. By Lemma[3.1.5| every vertex u in C; has fewer than
C;eNE(Cy)
t(u) neighbours in S if and only if

(@i— D+ D a<tC)

C;eNu(Cy)
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The next lemma follows readily from the three lemmas above and the definition of the

sequence (z1,Tg,...,2q) and harmless set.

Lemma 3.1.6. Let G = (V, E) be a graph such that V' can be partitioned into at most d
type classes C, ..., Cy. The sequence (x1, s, ..., x4) represents a harmless set S of G if and

only if (x1,z9,...,z4) satisfies

1. z; € {0,1,...,|C;|]} fori=1,2,...,d

2. Y oz <t(Cy) forall C; € T.
C;eN(C)

3.+ >, x; <t(Cy) and z; < o(C;) for all C; € Co
C;eNE(Cy)

4. (l’l — 1) -+ Z X < t(CZ> and Oé(Cl) S Z; S |Cz| for all CZ S Cz.
CjGNH(Ci)

In the following, we present an ILP formulation for HARMLESS SET parameterized by neigh-
bourhood diversity for a given guess:

Maximize T;
C;
Subject to
x; €{0,1,...,|Ci|} fori=1,2,...,d

Z z; < t(C;), forall C; €T,

CjENH(Ci)
T + Z x; < t(C;) and z; < a(C;) for all C; € Co
C;eNu(C)
(@i =1+ Y a; <t(Ci) and a(Cy) < x; < |Cy| for all C; € Cx
C’jENH(CZ‘)

Example 3. Consider a graph composed of a clique C' of size 5 plus a vertex u adjacent to
a vertex v of the clique as shown in Figure We set unanimity thresholds, so t(u) = 1,
t(v) = 5 and t(x) = 4 for all z in C'\ {v}. The type classes are C; = {u}, Cy = {v} and
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Figure 3.4: The graph in Example

C3 = C\ {v}. Here a(Cy) =1, a(Cs) = 1 and «o(C3) = |C3| = 4. We guess C1,Cy € T and
C3 € C>. Then we end up with the following ILP:

max X1+ Tg9+ T3
st. xy <1
T1+x3<95

r3— 1+ 29 <4 and x3 =4

Note that x5 < 1 implies that zo = 0; 21 + 23 < 5 and x3 = 4 imply x; = 0. It is easy to see
that ©; = 9 = 0, x3 = 4 is an optimal solution and represent a valid harmless set for the

graph.

Running time: In our formulation for HARMLESS SET, we have at most d variables.
The value of the objective function is bounded by n and the value of any variable in the
integer linear programming is also bounded by n. The constraints can be represented using
O(d?logn) bits. There are at most 2¢ guesses, and due to Lemma|2.3.2| each ILP formula
for a given guess can be solved in time d°@ . n®®) Thus Theorem holds.

3.1.2 HARMLESS SET Parameterized by Twin Cover

In this section, we present an FPT algorithm for HARMLESS SET parameterized by twin-

cover. That is, we prove the following theorem:

o

Theorem 3.1.7. HARMLESS SET can be solved in time 20 2%) . nOW) where k is the twin-

cover of the input graph.

Outline of the algorithm. Given an n-vertex graph G with tc(G) < k, we first find a twin

cover X of size at most k. We next guess Sx = S N X where S is a largest harmless set
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in G. Define S¢ = V \ S. There are at most 2* candidates for Sx as each member of X
has two options: either in S N X or SN X. Finally we reduce the problem of finding the
rest of S to an integer linear programming (ILP) optimization with at most 2% variables.
Again, using ILP optimization which is fixed-parameter tractable when parameterized by
the number of variables [47], we can conclude that our problem is fixed-parameter tractable

when parameterized by the twin-cover number.

Characterizations of a harmless set S with a twin-cover X. Let G = (V, E) be a graph and
X C V be a twin-cover of G. Then C = G — X is a collection of disjoint cliques, that is
C ={C4, s, ...}. For the graph in Figure[3.5] we have X = {v1,v,} and the disjoint cliques
are C1 = {vs,v4}, Co = {vs} and C3 = {vg,v7,v3}. The threshold t(C;) of a clique C; is

defined similar to the previous section to be
t(C;) = min{t(v) | v € C;}.

Let a(C;) be the number of vertices in C; with threshold value ¢(C;). It may be observed that
from a clique it is always better to include the vertices with lower thresholds in the solution.
If @ and b are true twins with ¢(a) < #(b) then any solution S containing b but not a can
be replaced with a solution containing a but not b. If a is in the solution but not b, then a
has one fewer neighbour in the solution compared to b. The fact that a has a lower degree
in the solution helps a to satisfy the required threshold condition as t(a) < t(b). We define
C-={CeC : afC)>t(C)—|Ns(O)|} and C< = {C € C : a(C) < t(C) — |Ns, (C)|}
where Ng, (C) denotes the neighbourhood of C' in Sx, that is, the set of all vertices in Sx
adjacent to all elements of C'. A clique C' € C is said to be S-good if every vertex u € C has

fewer than ¢(u) neighbours in S.

Lemma 3.1.8. Assume that C' is in C~. Then C is S-good if and only if |S N C| <
HO) — [Nsy (C)] — 1.

Proof. Suppose C' is S-good and suppose for the sake of contradiction, that [S N C| >
t(C) — |Ns (C)]. TSN C| =t(C) — |Ng, (C)] then |SNC| < a(C). This implies there
exists a vertex ug € S°N C with t(up) = t(C). Note that ds(ug) = |S N C| + |Ng, (C)| =
t(C) = t(up), a contradiction to the assumption that C' is S-good. Let us assume that
1SN C| > t(C) — [Nsy (C)] + 1. Let u be an arbitrary vertex in C'. Then dg(u) > |S N
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C| — 1+ |Ns, (C)] > t(C) > t(u), which is again a contradiction. This proves the forward
direction.

On the other hand, let us assume that |[SNC| < t(C')—|Ng, (C)|—1. It implies that |[SNC| <
a(C) and this ensures that there exists at least one vertex g in SN C with threshold ¢(C).
By Lemma it is enough to check whether vy € S°NC with threshold ¢(C') satisfies the
threshold condition. Clearly, ds(ug) = [SNC|+|Ngy (C)] < t(C)—|Nsy (C)|—1+|Ns, (C)| =
t(C) — 1 satisfies the threshold condition. Therefore, C' is S-good. O

Lemma 3.1.9. Assume that C is in C<. Then C is S-good if and only if |S N C|] <
t(C) = [Ny (C)].

Proof. Suppose C'is S-good. Let ug € C' with threshold t(ug) = ¢(C). Then vy also has fewer
than ¢(ug) neighbours in S, that is, ds(ug) < |SNC| + Ng, (C) < t(ug) = t(C). Therefore,
we get |[SNC| <t(C) — |Ngy (C)].

On the other hand, first suppose that |S N C| = t(C) — |Ng,(C)|. Therefore, we can
say that |S N C| > a(C). It means that all the vertices with least threshold are inside the
solution. Let u be an arbitrary vertex in S N C with threshold ¢(u). By Lemma it
is enough to check whether u satisfies the threshold condition. It is easy to observe that
ds(u) = [SNC| =1+ |Ng (C)] =t(C) — 1 < t(C) = t(u), that is, u satisfies the threshold
condition. Now suppose |S N C| = t(C) — |Ng, (C)| — § for some integer 6 > 1. Take an
arbitrary vertex u € C. We have dg(u) < |SNC|+ |Ng (C)] = t(C) — 9 < t(C) < t(u).
This implies that C' is S-good. O

We partition the family C of cliques into twin classes Cy,Ca, . . ., C;, where t < 2%, Two cliques
C; and C; are in the same twin class if and only if they have the same neighbours in X, that
is, Nx(C;) = Nx(C}). For example, the graph G in Figure|3.5|has two twin classes C; and
Cy, where C; consists of cliques C] = {v3,v4}, Co = {vs} and Cy consists of C5 = {vg, v7, v3}.
For each twin class C;, we associate a variable x; that indicates the number of vertices from

C; that are in the solution S, that is, z; = > |C'NS|. The variables z; determine S. The
CceC;
t

objective is to maximize ) x; under the conditions
i=1

0<m< Y (HO) = INs (O = 1)+ D (HC) - INsc(C)])

CceC-nNC; CECSQCZ‘
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and the additional conditions described below. The above constraints make sure that the
vertices of twin class C; satisfy the threshold condition. Next, we add k& more constraints
to make sure that all the vertices in X satisfy threshold condition. Let u € X be an ar-
bitrary vertex. We need dg(u) < t(u). Therefore, for each v € X, we add the constraint

ds(u) = dg, (u) + > x; < t(u). In the following, we present an ILP formula-
Ci : N(u)NC#0 V CeC;
tion for HARMLESS SET for a given Sx:

t
Maximize Z i, t < oF
i=1
Subject to
0z Y (HO) = INs (O =1)+ D (HO) = INsi(C)])  ¥C
CceC=NC; CECSﬁCi
ds(u) = dg, (u) + Z r <tlu) VYuelX
Ci : N(u)NC#£0 vV CeC;
Let (xq,2,...,2;) be an optimal solution of ILP for a given Sx where x; is the number

of vertices from the twin class C; that are in S. In order to obtain S, we need to choose
x; vertices from the cliques in C; for all .. We arbitrarily choose at most ¢(C') — |Ng, (C)]
vertices from the clique C' if C' € C< NC; and at most ¢(C') — | Ng, (C)| — 1 vertices from the
clique C'if C' € C- NC; so that the total number of vertices selected from C; is z;. Note that

such a selection is possible because of the constraint on x; in the ILP.

Running time: In our ILP formulation for HARMLESS SET parameterized by twin-cover,
we have at most 2F variables. The value of the objective function is bounded by n and the
value of any variable in the integer linear programming is also bounded by n. The constraints
can be represented using O(2* log n) bits. Lemma implies that we can solve the problem

(k2¥)

for a given Sy in time 2¢ n®M_ As there are at most 2¢ candidates for Sy, Theorem

holds.

Example 4. Consider the graph shown in Figure We set unanimity thresholds, so
t(vs) = 2, t(v2) = 6, and t(v1) = t(vs) = t(vg) = t(vg) = t(v7) = t(vg) = 3. Here X =
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{v1,v2} is a minimum size twin-cover and G \ X is the collection of disjoint cliques C =
{vs,v4}, Cy = {vs} and C3 = {vs,v7,vs}. These three cliques are partitioned into two
twin classes C1 and Cy where Cy consists of cliques C1, Cy and Co consists of C3. We have
t(Ch) = 3, t(Cy) =2, t(C5) = 3 and a(Cy) =2, «a(Cs) =1, a(Cs) = 3. Take Sx = {v1},

U3 U6
@) Q
U2
<K
O O
Us v7

Figure 3.5: The graph in Example[4]

that is, vy is in the solution. Given Sx = {v1}, we observe that Cy,Cy, Cy are in C< and we
end up with the following ILP:

maxr T+ X9

It is easy to see that x1 = 2, x9 = 3 is an optimal solution. As x1 = 2 we randomly choose
two wvertices say vy and vs from Cy and as xo = 3 we choose three vertices vg, v7,vg from
Cy. Thus xy = 2, xo = 3 represent a valid harmless set {vy, vs,vs, vg, v7,v8} of size 6 for the
graph. Similarly for Sx = {vs} and Sx = 0, we get valid harmless sets of size 5. It may be
noted that Sx = {vy,va} is not a valid choice as vy does not satisfy the threshold condition

if both v1 and vy are in the solution. Therefore G has a mazimum harmless set of size 6.

3.1.3 HARMLESS SET Parameterized by Vertex Integrity

In this section, we present an FPT algorithm for HARMLESS SET parameterized by vertex
integrity. This vulnerability measure was introduced by Barefoot et al. [7] in 1987. For an
overview of structural results on vertex integrity, we refer the reader to a survey on vertex

integrity by Bagga et al. |6].
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2
Theorem 3.1.10. HARMLESS SET can be solved in time 22°" . nO0) where k is the vertex

integrity of the input graph.

Outline of the algorithm. Let X be a k-vi-set of G. Such a set can be found in O(k*+1n)
time [37]. We next guess Sx = S N X where S is a largest harmless set in G. There are at
most 2¥ candidates for Sx as each member of X has two options: either in SN X or S¢N X.
Finally we reduce the problem of finding the rest of S to an integer linear programming

(ILP) optimization with number of variables depend only on k.

Characterizations of a harmless set S with a k-vi-set X. Let G = (V, E) be a graph and
X C V be a k-vi-set of G. Then C = G — X is a collection of disjoint components, that
is C = {C4,Cy, ...} such that | X| 4 |C;| < k for all i. We know C can be partitioned into
equivalent classes C1,Cs,.... Let C) be a representative of the equivalence class C; and let
v € C;. Note that v has neighbours only in X U V/(C)), that is, N(v) C X UV(C}). Suppose
the intersection of the solution S with X is Sx = S N X and the intersection of S with
the component Cj is A = SN C; C (). Therefore v € () satisfies the threshold condition
if dg(v) = |Ngy(v)] + |[Na(v)| < t(v). We say A C C) is a walid selection for Cj if every
vertex of ( satisfies the threshold condition when the vertices of AU Sx are in the solution.
Similarly, we say A C C is a valid selection for C' € C;, C # C, if every vertex of C satisfies
the threshold condition when the vertices of g(A) U Sx are in the solution, where g is an
isomorphism from G[X U V(C))] to G[X U V(C)] that fixes X. It is important to note that
given two connected component C; and C5 from the same equivalence class C;, a subset
A C () might be valid selection for one connected component but may not be valid for the

other connected component as the threshold values of vertices in C and Cy can differ.

Given Sx = SN X, for every equivalence class C;, we define the set of valid selection
VS(l) = {A C () | Ais a valid selection for some C' € Cl}.

We denote by p(A, 1) the number of components in C; where A is a valid selection. Similarly,
we denote by p(A;,, Ay, ..., A, 1) the number of components in C; where A;, A,,,..., A;,
are valid selection. Note that for each component there may be more than one valid se-
lections. But while forming the harmless set we can pick at most one valid selection for

every component. Observe that as long as we are taking exactly one valid selection for each
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Figure 3.6: The components Cy and C3 of G — X have the same (G, X)—type. That is,
C5 and (3 are in the same equivalence class. Here the four components are partitioned
into three equivalent classes C; = {C1}, Co = {C5,C53} and C3 = {C,}. Note that given
Sx = {vs,vg,v5}, A = {d} is a valid selection for Cy but g(A) = {f} is not a valid selection
for C5, thus p(A,Cs) = 1; B is not a valid selection for C; C' is a valid selection for Cj.

connected component, we are guaranteed that all the vertices in G — X satisfy the threshold
conditions. We need to add constraints in ILP separately to make sure that every vertex
of X also satisfies the threshold condition. Let x(A,[) denote the number of components in
C, for which A has been picked as a valid selection in the final solution. They satisfy the

following properties:

(A, 1) < p(A;, 1) forall A; € VS(I)
$<Az, l) + fL’(Aj, l) < p(AZ, l) + p(Aj, l) - p(Am Aj, l) for all Ai, Aj S VS(Z)

S a(4n)) =l

A;€VS(l)

Example 5. Suppose the equivalent class C; has three components Cy,Cs,C3. Suppose
Ay, Ay, As are wvalid selections for the component Cy; Ay, A3 are valid selections for the
component Cs, and Ay, Az are valid selections for the component Cs. Clearly, p(Ai,l) =
2, p(Ag,l) = 2, p(As3,1) = 3, p(A1, Az, 1) = 1, p(Ay, A3, l) = 2, p(Ag, As,l) = 2 and
p(A1, Ag, A3, 1) = 1. Then x(Aq, 1), x(As,l) and x(As,1) satisfy the following constraints:

1. l’(Al,l) S 2,$(A2,l) S 2,.’13'(A3,l) S 3

p(Al,l) + ,O(Ag,l) — p(Al,AQ,l) =2+2-1=3
p(Al,l) + p(Ag,l) - p(Al,A:g,l) =24+3-2=3

VARVAN
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ZL’(AQ, l) + .Z'(Ag, l) S p(AQ, l) + ,O(Ag, l) - p(AQ, A3, l) =2+3-2=3
3. w(Ayl) + x(Ag, 1) + 2(As,1) = |C| =3
Therefore the possible solutions are (x(Ay,l), x(As, 1), x(As,1)) = (0,0,3), (0,1,2), (0,2,1),
(1,1,1), (1,0,2), (1,2,0), (2,0,1), (2,1,0). Note that (0,0, 3) indicates As is valid selection

in three components Cy, Cy and Cs; similarly (0, 1,2) indicates As is a valid selection in one

component and Az is a valid selection in two components.

In the following, we present an ILP formulation for HARMLESS SET for a given Sx:

Maxnmzez Z |A| x z(A,l)

I Aevs(l
Subject to
(A1) < p(Al) VI&V A eVS(l)
o(Ai, 1) +2(A;, 1) < p(Ai 1) + p(A5,0) — p(A;, Ay l) VI&Y A A e VS(I)

Y owA)=lal VI

A;€VS(l)

> Z w)NAl xz(Al) <tlu) V weX

L Aevs(l

Running time: In our ILP formulation for HARMLESS SET parameterized by vertex in-
tegrity, the number of variables is bounded by 20(-*) " The reason is this. The number of
equivalence classes is bounded by 20(:) a5 each component of G — X has at most k vertices
and hence at most O(k?) edges. Also for each equivalence class the number of valid inter-
sections is bounded by 2* as choosing a subset of vertices fixes the intersection. This implies
that the number of variables is at most 2°**). The value of the objective function is bounded
by n and the value of any variable in the integer linear programming is also bounded by n.
The constraints can be represented using 20%*) -1og n bits. Lemmaimplies that we can

o)

2
solve the problem for a given Sx in time 9270 O There are at most 2* candidates for

Sx, thus Theorem |3.1.10[holds.
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3.2 W]1]-Hardness results

3.2.1 HARMLESS SET Parameterized by Treewidth

Bazgan and Chopin [10] proved that HARMLESS SET is FPT when parameterized by the
combined parameters treewidth and solution size. However, it remains open whether the
problem might belong to FPT when parameterized only by the treewidth of the input graph.
In this section we resolve this open problem by showing that HARMLESS SET is indeed W|[1]-
hard when parameterized by treewidth alone, even when restricted to bipartite graphs. This
result is also proved simultaneously and independently by Drange, Muzi and Reidl [38]. Our
proof is very different from their proof. First, we show that HARMLESS SET is W[1]|-hard
parameterized by a vertex deletion set to trees of height at most three, that is, a subset D
of the vertices of the graph such that every component in the graph, after removing D, is
a tree of height at most three. Clearly trees of height at most three are trivially acyclic.
It is easy to verify that such trees have pathwidth [93] at most three and hence treewidth
at most three, which implies that HARMLESS SET is W|[1|-hard when parameterized by the
treewidth of the input graph.

We show our hardness result for HARMLESS SET using reduction from MULTIDIMENSIONAL
RELAXED SUBSET SUM (MRSS).

MULTIDIMENSIONAL SUBSET SUM (MSS)

Input: An integer k, a set S = {s1,...,8,} of vectors with s; € N¥ for every i with
1 <i <n and a target vector g € N*.

Parameter: k&

Question: Is there a subset S” C S such that > s= g7
ses’

We consider a variant of MSS that we require in our proofs. In the MULTIDIMENSIONAL
RELAXED SUBSET SUM (MRSS) problem, an additional integer k' is given (which will be
part of the parameter) and we ask whether there is a subset S’ C S with |S’| < &k’ such that

> s > g. This variant can be formalized as follows:
ses’
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MULTIDIMENSIONAL RELAXED SUBSET SUM (MRSS)

Input: An integer k, a set S = {s1,...,5,} of vectors with s; € N¥ for every i with
1 <i < n, a target vector ¢ € N¥ and an integer &'.

Parameter: k + £’

Question: Is there a subset S" C S with |S’| < k' such that > s> g7

ses’

It is known that MRSS is W][1]-hard when parameterized by the combined parameter k+ &',
even if all integers in the input are given in unary [74]. In this section, we prove the following

theorem:

Theorem 3.2.1. HARMLESS SET is W/1/-hard when parameterized by the size of a vertex

deletion set into trees of height at most 3, even when restricted to bipartite graphs.

Proof. Let (k, k', S, g) be an instance of MRSS. From this we construct an instance (G, ¢, ) of
HARMLESS SET the following way. We introduce three types of vertices: necessary vertices,
forbidden vertices and normal vertices. We want to make sure that necessary vertices are
always inside every solution and forbidden vertices are always outside every solution; and a
normal vertex could be inside or outside the solution. For each vector s € S, we introduce
a tree T of height three. For s € S, max(s) is the value of the largest coordinate of s and
max(S) is maximum of max(s) values. The tree T consists of vertices V(1) = A*UB*U{c*}
where the vertices in A* = {af,...,a

Bs = {b5,...,b°

max

fnax(s)} and c¢® are normal vertices and the vertices in
(S)} are necessary vertices. Make ¢® adjacent to every vertex of B*. We
also make af adjacent to bf for all 1 < i < max(s). Next, we create a set U = {uq, ..., ux} of
k necessary vertices into GG. For each 1 <i < k and s € S, make u; adjacent to exactly s(7)
many vertices of A° arbitrarily. We introduce three cycles C1, Cy, C3 of length four where
V(C1) = {a1,az2,a3,a4}, V(Cy) = {b1, b, b3, b4} and V(C3) = {c1, ¢, c3,¢4}. We will prove
why the vertices of these three cycles are always outside every solution. We make a; adjacent

to every vertex of |J A®, make b; adjacent to every vertex of |J B® and make ¢; adjacent

ses ses
to every vertex of |J{c*}. This completes the construction of graph G. Note that G is a
s€S

bipartite graph with bipartition

Vi=UU{a1,as3,b, by, c1,c3}1 U U B®

seS
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Figure 3.7: The graph G in the proof of Theorem constructed from MRSS instance

= {(2,1),(1,1),(1,2)},9 = (3,3),k = 2,k = 2. The set S" = {(2,1),(1,2)} forms a
solution of MRSS instance and the set H = {uy, us} UB** U B*>U B* U A*2U{c*, ¢*} forms
a harmless set in G.

and
‘/2 = {a27 Gy, bla b37 Ca, C4} U U A® U {CS}.

ses
We observe that if we delete the set U U {ay, az, as, ay, b1, ba, b3, by, c1} of size k + 9 from G
then we are left with trees of height at most three. We define the threshold function as

follows:

(

d(u) ifue{b} U A°U{c}
ses
1 ifue {a27a’37a47b27 b37b4702)c37c4}
2 ifue |J B*
t(u) = seS

(n — k') - max(S) + 1 if u=a
K+1 fu=c¢
S s(i) —g(i) +1 ifu=uw;forall 1 <i<k

\ s€ES

We set r = k 4+ n - max(S) + (n — k') - max(S) + £’. It may be noted that the sets {c"}
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and A® play the role of complimentary sets, that is, only one of them can contribute to the
solution. If both {¢*} and A® contribute to the solution then a vertex in B*® will fail to satisfy
the threshold condition. Also we argue in the later part of the proof that either we have
{c*} CHand A*NH =0, or A*C H and {¢*} N H = () for all s € S. The intention is that
for each solution S’ C S of instance I we have a solution candidate H in I’ such that s € S’
entails {¢°} C H and A*N H =0, and s ¢ S’ entails A* C H and {¢*} N H = 0.

Now we show that our reduction is correct. That is, we claim (k, k', S, g) is a yes instance
of MRSS if and only if (G,t,r) is a yes instance of HARMLESS SET. Towards showing the

forward direction, let S C S be such that |S’| < k" and > s > g. We claim that the set
seS’

H=vulJB'Uu |J A ulJ{}

ses seS\S’ ses’

is a harmless set of size at least r. It is easy to see that |H| > r. Next, we show that all the
vertices in G satisfy the threshold condition. Let u be an arbitrary vertex of G. If u = w; is
an element of U, then d(u;) = > s(i) and > s > g. Hence, we get di(u;) < > s(i)—g(i) <

t(u;)) = > s(i) —g(i)+ 1. If uselg an elemgg’f/of U A%, then dy(u) = d(u) — iei d(u) = t(u)

as a, ¢ jLEIS Similarly, every vertex of |J {cs} sat?gées the threshold condition as ¢; ¢ H. For

each b} € |J B*, we have either a] orseckz inside the solution and by ¢ H. Thus dy(bf) =1 <

2 = t(bf).siigence all the vertices in US B¢ satisfy the threshold condition. As |S’| < k/, we
s€

see that ¢; has at most &’ neighbours inside H, thus dy(c1) = k¥ < k' +1 = t(¢;). For the

rest of the vertices in H it is easy to verify that the threshold condition is satisfied.

Towards showing the reverse direction of the claim, let H be a harmless set of size at least r
in G. We need the following three simple observations. First, H N{a;, b, c; | 1 <i <4} = ().
Otherwise one of the vertices of {a;,b;,¢; | 2 < i < 4} will fail to satisfy the threshold
condition. Therefore the vertices in {a;, b;,¢; | 1 < i < 4} are forbidden vertices. Second,
for each s € S, {¢°} and A® play the role of complimentary sets, that is, only one of them
can contribute to the solution H. If both {¢*} and A® contribute to H then some vertex
b in B* will have two neighbours in the solution but ¢(b) = 2. Therefore b will not satisfy
the threshold condition, a contradiction to the assumption that H is a harmless set. Third,

the vertices of U and |J B* are always inside every solution of size r. Because of the first
seS
two observations, it would not be possible to get a harmless set of size at least r unless we
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include all the vertices of U and |J B® in the solution. Clearly U and |J B*® can contribute
s€S seS
to the solution at most k and n - max(.S) vertices respectively. We also observe that the set

U A® can contribute at most (n — &’) - max(S) vertices to the solution due to the fact that
seS
t(ay) = (n — k') - max(S) + 1. Therefore, the only way to have a harmless set of size at least

r is that |J {c*} contributes at least &’ elements to H. Since t(c;) = k' + 1, the set | {c¢°}
s€S seS
can contribute at most k' elements to H. Therefore, the set |J{c*} contributes exactly &’
seS
elements to H. We define

S'={seS|ceH}
From here, we see that H = UU | B* |J A° |J {¢}. Since each u; € U satisfies the
s€S  seS\§'  sed
threshold condition, we have dy(w;) = > s(i) = > s(i) — > s(i) < t(uw;) = > s(i) —
seS\S’ ses ses’ ses
g(i) + 1. This implies that > s(i) > g¢(i) for all 1 < i < k. Therefore (k, k', S,g) is a

ses’
yes-instance. 0

Clearly trees of height at most three are trivially acyclic. Moreover, it is easy to verify
that such trees have pathwidth [93] and treedepth |110] at most three, which implies:

Theorem 3.2.2. HARMLESS SET is W/1/-hard when parameterized by any of the following
parameters:

e the feedback vertex set number,

e the pathwidth and hence also treewidth of the input graph,

e the size of a minimum set of vertices whose deletion results in components of path-

width /treedepth at most three,

even when restricted to bipartite graphs.

3.2.2 HARMLESS SET Parameterized by Cluster Vertex Deletion Num-
ber

We prove that HARMLESS SET is W[1]-hard when parameterized by the cluster vertex dele-

tion number of the input graph. The cluster vertex deletion number of a graph is the mini-
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mum number of its vertices whose deletion results in a disjoint union of complete graphs [34].
This generalizes the vertex cover number, provides an upper bound to the clique-width and
is related to the previously studied notion of the twin cover of the graph under consideration.
We show our hardness result for HARMLESS SET using reduction from MULTIDIMENSIONAL
RELAXED SUBSET SUM (MRSS). We prove the following theorem:

Theorem 3.2.3. HARMLESS SET is W/[I[-hard when parameterized by the cluster vertex

deletion number of the input graph.

Proof. Let (k,k',S,g) be an instance of MRSS. From this we construct an instance (G, t,r)
of HARMLESS SET the following way. We introduce two types of vertices: forbidden vertices
and normal vertices. We want to make sure that forbidden vertices are always outside every

solution and a normal vertex could be inside or outside the solution.

S1
a; 9

51
bi' o

Figure 3.8: The graph G in the proof of Theorem constructed from MRSS instance
S ={(21),(1,1),(1,2)},9 = (3,3),k = 2,k = 2. The set 5" = {(2,1),(1,2)} forms a
solution of MRSS instance and the set H = B** \ bi* U B* \ bj® U A*? forms a harmless set
in G.

For each vector s € S, we introduce a clique C*. For s € S, max(s) is the value
of the largest coordinate of s and max(S) is maximum of max(s) values. The clique C*
consists of normal vertices V(C*) = A*U B* where A* = {aj,...,a}, (5,1} and B® =
{01, -+ bpax(sy11 - We introduce a set of k forbidden vertices U = {ui, ..., ux} into G. For
each 1 <1i <k and s € S, make u; adjacent to exactly s(i) many vertices of A® arbitrarily.
Finally, we add two forbidden vertices  and y; make x adjacent to every vertex of U and y.

This completes the construction of graph G. Note that deletion of the set U of size k from G
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results in a disjoint union of complete graphs. We define the threshold function as follows:

(

1 if u e {z,y}
max(S) + 1 ifue |J A°
t(u) _ seS
max(S) + 2 ifue | B®
seS
Yos(i)—g(i)+1 ifu=w;forall 1 <i<k
\ SES

We set r = k' - max(S) + (n— k) - (max(S) +1). The vertices of UU{x,y} are always outside
every solution as t(x) = t(y) = 1. Therefore the solution can take vertices only from n
cliques. We shall observe that |H N (A*U B*)| < max(S)+ 1 and this bound can be achieved
if and only if H N (A*U B®) = A°.

Now we show that our reduction is correct. That is, we claim (k, &/, S, g) is a yes-instance
of MRSS if and only if (G,t,r) is a yes-instance of HARMLESS SET. Towards showing the

forward direction, let S C S be such that |S’| < k" and > s > g. We claim that
ses’

H=]JB\{pi}u [J 4°

s/ s€S\ S/

is a harmless set in G of size at least r. Clearly |H| > r. Next, we show that each
vertex of G satisfies the threshold condition. Since x and y have no neighbours in H and

t(z) = t(y) = 1, they satisfy the threshold condition. Since each vertex of |J C* has at
seS’
most max(S) neighbours in H and has threshold value max(S) + 1 or max(.S) + 2, therefore

each vertex of |J C¥ satisfies the threshold condition. Every vertex of |J A® has exactly
s€s' seS\ S’
max(S) neighbours in H and has threshold value max(S) + 1; every vertex of |J B?® has
seS\S/
exactly max(S) 4+ 1 neighbours in H and has threshold value max(S) + 2. Therefore, every

vertex of |J C° satisfies the threshold condition. Let u; be an arbitrary element in U. As
s€S\ S’

2, 8(1) 2 g(i), we get du(w) = > (i) = > s(i) = 2 s(1) < 3 s(0) — g(i) < t(w).

ses’ seS\S’ seS ses’ seS

Towards showing the reverse direction, let H be a harmless set of size at least r in G.
It may be noted that H N (U U {x,y}) = 0, otherwise one of the vertices in {x,y} will
fail to satisfy the threshold condition. Observe that any clique C* can contribute at most

max(S) + 1 vertices, otherwise vertices of A® will fail to satisfy the threshold condition. We
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now prove the following simple claim.

Claim 3.2.1. If |C° N H| = max(S) + 1 then C°* N H = A°.

Proof. Targeting a contradiction, suppose |C* N H| = max(S) + 1 but C* N H # A®. That
is, there exists a vertex a® € A® such that «® ¢ H. As |C* N H| = max(S) + 1, we have

dy(a®) = max(S) + 1 = t(a®), which is a contradiction. This proves the claim. O

Note that n cliques together contribute at least r = &’ - max(S) + (n — k) - (max(S) + 1)
vertices to H and each clique can contribute at most max(S) + 1 vertices. Therefore, by
Pigeonhole principle, there are at least (n — k') cliques C® for which |H N C®| = max(S) + 1.
By the above claim, there are at least (n — k') cliques C* for which H N C* = A®. We define

S ={seS|HNC*# A},

Clearly for s € S’, we have |C* N H| < max(S). We construct H' from H as follows:

H =(H\|]JA)ul] B\ {5}

seS’ s€S’
Clearly |H'| > |H| and H' is a harmless set. Every vertex of |J C* satisfies the threshold
ses’
condition because every vertex of (J C* has max(S) neighbours in H and has threshold

seS’
value max(S) + 1. For each w; € U, we see that dpy/(u;) < dg(u;) < t(u;). For rest of the

vertices, we can easily verify that the threshold conditions are satisfied. This implies that

H' is a harmless set of size at least r. So we consider the harmless set to be of the form

H = |U A° | B*\ {bj}. Since each u; € U satisfies the threshold condition, we have
seS\S'  ses

dp(u;) = > s(i) = > s(i) — > s(i) < t(u;) = D s(i) — g(i) + 1. This implies that
seS\S’ ses ses’ seS

> s(i) > g(i) for 1 <i < k. Therefore (k, k', S, g) is a yes-instance. O
ses’

3.3 XP algorithm parameterized by clique-width

This section presents an XP-time algorithm for HARMLESS SET parameterized by clique-
width.
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Theorem 3.3.1. Given an n-vertex graph G and an irredundant c-expression T of G, HARM-
LESS SET is solvable in O(cn*®) time.

For each node t in a c-expression T, let G; be the vertex-labeled graph represented
by t. We denote by V; the vertex set of G;. For each label 7, we denote the set of i-
vertices in G; by V/'. For each node t in T, we construct a table dp;(r,s) € {true, false}
where r = (r(1),...,r(c)) and s = (s(1),...,s(c)) are c-dimensional vectors; for each index
i€{l1,2,...,c}, r(i) can take value from the set {0,...,n} and s(7) can take value from the
set {—n+1,...,n— 1} U{oco}. We set dp;(r, s) = true if and only if there exists a set S in
V; such that for all i € {1,2,..., ¢}

o (1) = |SNV;

o s(i) = minvevti{t(v) ~ |Ng,(v) N 5|}, with s(i) = oo if V = 0.
That is, r(i) denotes the number of the i-vertices in S and s(i) is the “surplus" at the weakest
t-vertex in S.

Let 7 be the root of the c-expression 1" of G. Then G has a harmless set of size h if there
exist r, s satisfying

1. dp.(r,s) = true;

2.3 r(i)=nh

3. min{s(i)} > 1.

In the following, we compute all entries dp;(r,s) in a bottom-up manner. There are
(n+1)°- (2n)¢ = O(n*) possible tuples (r,s). Thus, to prove Theorem [3.3.1] it is enough to

prove that each entry dp;(r,s) can be computed in time O(cn*¢) assuming that the entries

for the children of ¢ are already computed.

Lemma 3.3.2. For a leaf node ¢t with label i, dp;(r, s) can be computed in O(c) time.

Proof. Observe that dp;(r,s) = true if and only if r(j) = 0, s(j) = oo for all j # i, and

either
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The first case corresponds to S = (), and the second case corresponds to S = V;’. These

conditions can be checked in O(c) time.

Lemma 3.3.3. For a & node ¢, dp;(r, s) can be computed in O(cn?*®) time.

Proof. Let t; and t5 be the children of ¢ in 7. Then dp;(r, s) = true if and only if there exist
(r1,81) and (ry, sp) such that dpy, (ry,s1) = true, dp, (T2, 82) = true, r(i) = r1(i) + ra2(d),
and s(i) = min{sl(i), sz(z')} for all <. The number of possible pairs for (ry, r,) is (n+ 1) as
r, is uniquely determined by r;. There are at most (2n)¢ possible pairs for (sy, s5) as either
s1(1) = s(i) or s5(i) = s(i) for all 7. In total, there are (n + 1) - (2n)¢ = O(n*®) candidates.
Each candidate can be checked in O(c) time. Thus the lemma holds.

Lemma 3.3.4. For a 7, ; node t, dp(r, s) can be computed in O(c) time.

Proof. Let t' be the child of ¢ in T. Then, dp,(r,s) = true if and only if dpy(r,s’) = true

for some s’ with the following conditions:

e s(h) =s'(h) hold for all h € {1,2,...,c}\ {i,j};

o s(i) = s'(i) —r(j) and s(j) = s'(j) — x(i).
We now explain the condition for s(i). Recall that 7" is irredundant. That is, the graph Gy
does not have any edge between the i-vertices and the j-vertices. In Gy, an i-vertex has
exactly r(j) more neighbours in S and similarly a j-vertex has exactly r(i) more neighbours

in S. Thus we have s(i) = s'(i) — r(j) and s(j) = s'(j) — r(é). The lemma holds as there is

only one candidate for each s/(7) and s'(j).

Lemma 3.3.5. For a p;_,; node t, dp(r, s) can be computed in O(cn?) time.

Proof. Let t' be the child of ¢ in T.. Then, dp;(r,s) = true if and only if there exist r’, s’

such that dpy(r',s’) = true, where :

e r(i)=0,r(j) =r'(2) +1'(j), and r(h) =r'(h) for all h € {1,2,... ¢} \ {i,7};
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e s(i) = o0, s(j) = min{s'(i),s'(j)}, and s(h) = s'(h) for all h € {1,2,...,c}\ {i,j};

The number of possible pairs for (r'(),r'(j)) is O(n) as r'(j) is uniquely determined by r’(7).
There are at most O(n) possible pairs for (s/(i),s’(j)). In total, there are O(n?) candidates.
Each candidate can be checked in O(c) time, thus the lemma holds.

3.4 Closing Remarks and Future directions

Our results close a wide gap in the understanding of the complexity landscape of HARMLESS
SET parameterized by structural parameters. We have shown that HARMLESS SET is W|[1]-
hard parameterized by a wide range of fairly restrictive structural parameters such as the
feedback vertex set number, pathwidth, treedepth, and cluster vertex deletion number of the
input graph. On the positive side, we have given FPT algorithms when parameterized by
any of the following parameters: vertex integrity, neighbourhood diversity and twin cover.
To give an upper bound on the complexity, we give an XP-algorithm parameterized by
clique-width. The ﬁgure provides a summary of results.

It is interesting to figure out the parameterized complexity of HARMLESS SET with
respect to the parameters such as vertex deletion to disjoint paths and modular width.
Also it will be inetresting to know the exact class of these problems in W-hierarchy. For
MAJORITY HARMLESS SET, it will be interesting to see if the parameters such as treedepth,
feedback vertex set and cluster vertex deletion set allow FPT algorithms or the problem still

remains intractable.
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Figure 3.9: This is an overview of the parameterized complexity landscape for the HARM-
LESS SET problem with general thresholds. The stars highlight parameters that are covered
in this study. The T symbol highlight parameters that are simultaneously and independently
proved by Drange, Muzi and Reidl [38]. The problem is FPT parameterized by blue colored
parameters and W[1]-hard when parameterized by red colored parameters. The problem can
be solved in XP-time when parameterized by clique-width; hence the problem is in XP when
parameterized by each of the following parameters: tw, pw, fvs, td and cvd. The problem
remains unsettled when parameterized by mw.
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Chapter 4

Defensive Alliance in Graphs

A set S of vertices of a graph is a defensive alliance if, for each element of S, the majority of its
neighbours are in S. In this chapter, we study the parameterized complexity of DEFENSIVE
ALLIANCE, where the aim is to find a minimum size defensive alliance. Our main results
are the following: (1) DEFENSIVE ALLIANCE has been studied extensively during the last
twenty years, but the question whether it is FPT when parameterized by feedback vertex
set has still remained open. We prove that the problem is W|[1|-hard parameterized by a
wide range of fairly restrictive structural parameters such as the feedback vertex set number,
treewidth, pathwidth, and treedepth of the input graph; (2) the problem parameterized by
the vertex cover number of the input graph does not admit a polynomial compression unless
coNP C NP /poly, (3) it does not admit 2°™ algorithm under ETH, and (4) DEFENSIVE
ALLIANCE on circle graphs is NP-complete. This chapter is based on the papers [61}[72].

4.1 Hardness Results of Defensive Alliance

In this section we show that DEFENSIVE ALLIANCE is W[1]-hard when parameterized by
the size of a vertex deletion set into collection of stars, i.e., the size of a subset D of the
vertices of the graph such that every component in the graph, after removing D, is a star.
To show W|[1]-hardness of DEFENSIVE ALLIANCE, we reduce from the following problem:

71



MULTIDIMENSIONAL RELAXED SUBSET SUM (MRSS)

Input: An integer k, a set S = {s1,...,5,} of vectors with s; € N¥ for every i with
1 <i < n, a target vector t € N*¥ and an integer k’.

Parameter: k + £’

Question: Is there a subset S" C S with |S’| < k' such that > s > t?
ses’

It is known that MRSS is W|[1]-hard when parameterized by the combined parameter k+ &',
even if all integers in the input are given in unary [74]. To prove W|1]|-hardness of DEFENSIVE
ALLIANCE, we reduce MRSS to DEFENSIVE ALLIANCE.

Construction: Let I = (k, k', S,t) be an instance of MRSS. From this we construct an
instance I’ = (G,r) of DEFENSIVE ALLIANCE the following way. See Figure for an
illustration. Before we formally define our reduction, we briefly describe the intuition. We
introduce three types of vertices: necessary vertices, forbidden vertices and normal vertices.
We often indicate necessary vertices by means of a triangular node shape, and forbidden
vertices by means of a square node shape, and normal vertices by means of a circular node
shape. We want to make sure that necessary vertices are always inside every solution and
forbidden vertices are always outside every solution; and a normal vertex could be inside or
outside the solution. Note that it is easy to force a forbidden vertex outside every solution
by simply increasing its degree in the graph. Specifically, if we are looking for a defensive
alliance of size at most r then vertices of degree more than 2r are always outside every
solution. The challenging part is to force inclusion of all necessary vertices in every solution.
Our strategy is to first make sure that if even one necessary vertex is included in a solution
then that will force inclusion of all necessary vertices in the solution. In order to do this, we
have introduced a “chain" like gadget consisting of some necessary vertices. Next, in order
to force inclusion of at least one necessary vertex in every solution, we consider protection
of normal vertices. For all normal vertices, we add some forbidden and necessary neighbours

such that their protection will require at least one necessary vertex.

Let s = (s(1),s(2),...,s(k)) € S and let max(s) denote the value of the largest coordinate

of s. Set N = > (2max(s) + 2). The vertex set of the constructed graph G is defined as
s€S
follows:

1. We introduce a set of k necessary vertices U = {uy,us,...,ux}. For every u; € U,
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A‘A@ m'!,//,”% A
LANDS W

MRSS instance

(3,3), k = 2 and k' = 2. The vertices ¢t and t, and their

adjacency are not shown. Every square vertex is adjacent to 2r + 2 vertices which are also

not shown in the diagram.

Figure 4.1: The graph G in the proof of Theorem [4.1.4] constructed for

S ={(2,1),(1,1),(1,2)}, t

+ 2t(i) forbidden vertices.

(4)

S

-2
seS

create a set VDi of 2N

}. For every f € F, create

{f17f27f3

2. We introduce a set of three necessary vertices F

a set VfD of 2N forbidden vertices.

Let

f27f37u1‘

'7uk7f1a

Uy, ..

3. Consider the vertices of U U F' in the following order:

o (ug—1, ug), (uk, f1), (f1, f2), (f2, f3), (fs,u1)} be the set of pairs of

(ul,u2), ..

consecutive vertices.

P

For each pair (x,y) € P, create a set of N necessary vertices

aset V

of N forbidden vertices, and a special necessary vertex

O
Yy

)

}

N
LR

{hyy, -

H,, =

0
zy*

h

RS, hS'}

= {hD7

4. We introduce a set of three forbidden vertices H-
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5. For each vector s € S, we introduce two stars. The first star has internal node x, and

max(s) + 1 leaves A; = {ai,...,ap, (511 }; the second star has internal node y, and
max(s) + 1 leaves By = {bf, ..., b}, ()11}
6. We introduce a set V,” = {a7, ..., ag, s} of k + 5 forbidden vertices.

We now create the edge set of G.

1. For every u; € U, make u; adjacent to every vertex of VE.
2. For every f € F', make f adjacent to every vertex of VfD.

3. For each pair (z,y) € P, make x and y adjacent to every vertex of H,,; make hgy

adjacent to every vertex of H,, U ny
4. For each pair (r,y) € P, make every vertex of H,, adjacent to every vertex of H".

5. For each u; € U and for each s € S, we make u; adjacent to exactly s(i) many vertices

of A in an arbitrary manner. Make every vertex of I’ adjacent to every vertex of
U As U B,.

seS

6. For each s € S, we make each a® € A, adjacent to exactly |Ny(a®)| + 5 many vertices

of VI arbitrarily.

We define the set of necessary vertices Vo = UUFU |J Hg,U{h),}, the set of forbidden
(z,y)eP
vertices Vo = V2 UHYU U VU UV, U U V7 and r = (k+3)N + 2k +6 +
(z,y)EP uel feF

> (max(s) + 1) + k. For every x € Vp, create a set V, of 2r + 2 vertices, and make z
seS
adjacent to every vertex of V. We also add two vertices ¢ and t’. The newly added vertices

t, t' and the vertices in V, are not shown in Figure Make ¢ adjacent to every vertex

of V; forx e U V5, U UV, U U V' and make t' adjacent to every vertex of V, for
(z,y)EP uelU feF

x € VF'U HY. This completes the construction of I

The proof of Lemma [4.1.3] explains how inclusion of one vertex from U U F' ensures
inclusion of all necessary vertices Va in the solution. In order to make sure that at least
one necessary vertex from V is included in every solution, we consider protection of normal

vertices. For each normal vertex, we add some forbidden and necessary neighbours such
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that its protection requires at least one necessary vertex. As all the vertices in V5 are inside
every solution, the idea in the remaining part of the reduction is hidden in the protection of
vertices in U. Note that a normal vertex may or may not be included in the solution. The
vertices of two star graphs that we have introduced corresponding to each vector in S are
normal vertices. Note that the vertices in U have neighbours only in star graphs containing
Ag’s. Now, the construction makes sure that if a defensive alliance uses a vertex from A,
then it must pay a cost of one extra vertex x4 by forcing it to be inside the solution which
is not true for B,. As this is a minimization problem, every defensive alliance uses either A;
for protection of vertices in U and pay a cost of one extra vertex or uses Bs. At the end, we
argue that if the defensive alliance includes A, then we add the corresponding vector s to
the solution of MRSS, otherwise not.

Lemma 4.1.1. The size of a vertex deletion set of G into collection of stars, is 3k + 16.

Moreover, G is a bipartite graph.

Proof. Observe that if we remove the set UUF U H" UV U{hY, | (z,y) € P} U{t,t'} of
3k 4 16 vertices from G then we are left with only star graphs. Moreover, GG is a bipartite
graph with bipartition

vi=H vV uvuru{ry | vau U ) ulU{esud

zeVR\(HBUVD) (z,y)EP seS

and

w={tyu |J vyuH,)ulJvvulJviu |J v.ulJAuB,).

(z,y)EP uelU fer xEHDUVaD seS

]

Lemma 4.1.2. If (k, k', S,t) is a positive instance of MRSS then G has a defensive alliance

of size at most r.

Proof. Let S" C S be such that |S’| < k" and > s >t. We claim that the set
sesS’

R=UUFU |J Hyu{n}ul]JAu{z}u |J B,

(z,y)eP ses’ seS\S’
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is a defensive alliance in G such that |R| < r. Let x be an arbitrary element of R.
Case 1: If x = u; € U, then the neighbours of u; in R are the elements in H,, ,,, U Hy,qy,.,
and s(7) elements of A; if s € S’. Thus

dp(u;) = s(i) + 2N,

ses’

The neighbours of u; in R® are the elements of V) and s(¢) elements of A, if s € S\ S". Thus

dre(u) = Y s(@)+[Vil= Y s(i)+2N = s(i)+2t(i

seS\S' seS\S’ s€S
-~

size of Ve

Note that

dre(u) = 2N + Y s(i) =Y s(i) + 2t(i)

s€S\S' s€S
= = (s~ 3 s)) + 20
sES seS\S’
=2N — Y s(i) + 2t(4)
seS’
=2N + > (i) +2 (t(i) - s(i))
ses’ s€S5’

Therefore, we have dg(u;) + 1 > dge(u;), and hence w; is protected.

Case 2: If v = a® € A,, then dgr(a®) = |Ny(a®)| + |{f1, fo, f3,2s}| = |Nu(a®)| + 4 and
dge(a®) = |Ny(a®)| + 5. Therefore, we get dg(a®) + 1 > dge(a®).

Case 3: lf v = f; € F, then Ng(fi) = U AsU U BsUHy,p UHyy, and Nge(fi) =
s€S" s€5\S"

U BsU U AsUVE. As |A| = |B|, |[Huyp, | = |Hpp,| = N and V| = 2N + 1, we have

€S’ seS\S'

dr(f1) +1 > dge(f1). We can similarly check that {f,, fo} are also protected.

For the rest of the vertices in R, it is easy to see that dg(z) + 1 > dge(z). Therefore, (G,r)
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is a yes-instance of DEFENSIVE ALLIANCE. O

Lemma 4.1.3. If G has a defensive alliance of size at most v then (k, k', S,t) is a positive
nstance of MRSS.

Proof. Let R be a defensive alliance in G of size at most r. It is easy to see that (VoU{t,t'})N
R = () as any defensive alliance of size at most r cannot contain vertices of degree greater than

2r. This also shows that (J V, N R = 0. Now we show that Vo = UUF |J HgU{hJ,} C
zeVQ ((E,y)EP

R. We claim that if VA N R # 0 then VA C R.

Case 1: Suppose R contains u; from U. We observe that if some hy,,,, € Hy, .y, is not in R

then A . is also not in R. This implies that no vertex from H,,,, is in R. Since the elements

of Hyu, UV, are not in R, we get dge(u1) > [Hypup| + V)| = N +2N — Zs(l) + 2t(1).

sesS

-~

J/

size of vl
On the other hand the elements of Hy,,, and s(1) nodes from A, for each s € S could be
in R. Thus dr(u;) < N+ > s(1). This implies that u; is not protected in R which is

ses
a contradiction as u; € R. This implies that Hy,,,, U {hS

argument for hy, ,, € Hyyy,, we see that Hy,, U{h% , } C R. Clearly, this shows that f;

faur
and us are in R for protection of vertices in H,,y, U Hy,,,. Applying the same argument for

uy and f3, we get Hyyy, U{RY } C R and Hy,,, U {hY, } C R, respectively. Repeatedly

uu3 faux

applying the above argument, we get VA C R. Observe that this argument can be easily ex-
tended to all the vertices of U and F'. Therefore, we see that if (UUF)NR # (0, then VA C R.

} € R. Applying the same

Case 2: Suppose R contains h,, from H,, for some (x,y) € P. Clearly, this implies R
contains both x and y from U U F. Using Case 1, we get VAo C R.

Case 3: Suppose R contains h), for some (z,y) € P. Clearly, this implies that H,, C R.

Using Case 2, we get VAo C R.

Therefore we proved that if VA N R # @ then VAo C R. Next we claim that if R is non-
empty then R contains the set V. Since R is non-empty, we see that R must contain a

vertex from graph G. We consider the following cases:

Case 1: Suppose R contains a® from A for some s € S. Then we know that dgc(a®) >
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|Nuy(a®)] + 5. We see that a® is protected if and only if F N R # (. This implies that
VA N R # () which implies VA C R.

Case 2: Suppose R contains z, for some s € S. We know that =, has at least two neighbours
in Ay as max(s) + 1 > 2. This implies that z, is protected if and only if at least one vertex
a® € Ay is in R. Now, Case 1 implies that VAo C R.

Case 3: Suppose R contains b® € By for some s € S. Then we know that N(b%) = {ys} U F.
Clearly, the protection of b* requires at least one vertex from F. This implies that FNR # ().
Therefore, we have VAo N R # () and hence VA C R.

Case 4: Suppose R contains y, for some s € S. We know that y, has at least two neighbours
in B as max(s) + 1 > 2. This implies that y, is protected if and only if at least one vertex
b® € By is in R. Now, Case 3 implies that Vo C R.

This shows if R is non-empty then Vo C R. We know Vx contains exactly (k+3)N 42k +6

many vertices; thus besides the vertices of Va, there are at most ) (max(s) + 1) + &’ many

s€S
vertices in . Since f; € Rand dy, (fi) = dv;(f1) = 2N, it must have at least ) (max(s)+1)
s€S
many neighbours in R from the set |J As U Bs. We also observe that if a vertex a® from the

seS
set A, is in the solution then x also lie in the solution for the protection of a®. This shows

that at most &’ many sets of the form A, contribute to the solution as otherwise the size of
solution exceeds r. Therefore, any arbitrary defensive alliance R of size at most r can be

transformed to another defensive alliance R’ of size at most r as follows:

R=vy|JAu{z} |J B.

TsER zs€V(G)\R

We define a subset S’ = {3 €S |as€ R’}. Clearly, |S’| < k’. We claim that > s(i) > (1)

ses’
for all 1 < ¢ < k. Assume for the sake of contradiction that > s(i) < t(i) for some
seS’
i€{1,2,...,k}. Note that

ses’
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and

dre(us) = > (i) + V) = > s(i)+2N =) s(i) + 2t(i)

Then, we have

() = 2N = (Y s()) = D s0)) +2t(0)

ses s€S\ S
— 92N — Z s(1) + 2t(4)
=2N + Z s(i) + 2 <t(l) - Z 5@))

(.

>0 by assumption

We also know that u; € R’, which is a contradiction to the fact that R’ is a defensive al-
liance. Therefore, » s(i) > (i) for all 1 < ¢ < k. This shows that I = (k,k’,S,t) is a

ses’!
yes-nstance. OJ

We now prove our main theorem in this section:

Theorem 4.1.4. DEFENSIVE ALLIANCE is W|[1]-hard when parameterized by the size of a

vertex deletion set into collection of stars, even when restricted to bipartite graphs.

Proof. Given an instance (k,k’, S,t) of RBDS, we use the above construction to create an
instance (G, r) of DEFENSIVE ALLIANCE parameterized by the size of a vertex deletion set
into collection of stars. Lemmaand Lemmashow the correctness of our reduction,
while Lemma provides the bound on the size of a vertex deletion set into collection of
stars, showing that our new parameter is linearly bounded by the original parameter k. This

completes the proof. n

Clearly stars are trivially acyclic. Moreover, it is easy to verify that stars have pathwidth
[93] and treedepth [110] at most two, which implies:

Theorem 4.1.5. The DEFENSIVE ALLIANCE problem is W[1]-hard when parameterized by
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any of the following parameters:

e the feedback vertex set number,
e the treewidth and clique width of the input graph,

e the pathwidth and treedepth of the input graph,

even when restricted to bipartite graphs.

4.2 No Polynomial Kernel Parameterized by Vertex Cover

Number

A set C' C V is a vertex cover of G = (V| E) if each edge e € E has at least one endpoint
in X. The minimum size of a vertex cover in G is the vertex cover number of G, denoted by
ve(G). Parameterized by vertex cover number ve, DEFENSIVE ALLIANCE is FPT [92] and
in this section we prove the following kernelization hardness of DEFENSIVE ALLIANCE.

Theorem 4.2.1. DEFENSIVE ALLIANCE parameterized by the vertex cover number of the

input graph does not admit a polynomial compression unless coNP C NP /poly.

To prove Theorem we give a polynomial parameter transformation (PPT) from the
well-known RED BLUE DOMINATING SET problem (RBDS) to DEFENSIVE ALLIANCE pa-
rameterized by vertex cover number. Recall that in RBDS we are given a bipartite graph
G = (T'US, E) and an integer k, and we are asked whether there exists a vertex set X C S
of size at most k such that every vertex in T has at least one neighbour in X. We also refer
to the vertices of T' as terminals and to the vertices of S as sources or nonterminals. The

following theorem is known:

Theorem 4.2.2. [55] RBDS parameterized by |T'| does not admit a polynomial compression
unless coNP C NP /poly.
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4.2.1 Proof of Theorem [4.2.1]

By Theorem RBDS parameterized by |T'| does not admit a polynomial compression
unless coNP C NP /poly. To prove Theorem we give a PPT from RBDS parameterized
by |T| to DEFENSIVE ALLIANCE parameterized by the vertex cover number. Given an
instance (G = (T'U S, E), k) of RBDS, we construct an instance (G’,k’) of DEFENSIVE
ALLIANCE as follows. Take three distinct copies Ty, T1, T, of T, and let ¢; be the copy of
t € T in T;. Similarly, take two distinct copies Sy, S; of S, and let s; be the copy of s € S in
S;. Now for every vertex t € T} UT5, we introduce a set V; = {t1, ...t} of vertices and make
them adjacent to t where the number ¢ is defined later in the proof. Moreover, create three

vertices a, b and ¢ ans make them adjacent to every vertex of |J V;. We also make a and
teT1UTy

b adjacent to every vertex of Tp; and make a adjacent to every vertex of S;. If (¢,s) € E(G)
then we add the edges (to, o), (fo, 51), (1, $1) and (tq, so) in E(G’). Finally, we add a vertex
x* which is adjacent to every vertex of S; and also adjacent to exactly |S| many arbitrary
vertices from V; for some t € Ty UTy. We set k' = |T'| + |S| + k+ 1 and [ = 2k" + 1. Clearly
(G', k") can be computed in polynomial time. We observe that C' = Ty UTy UTyU{a, b, c, z*}
is a vertex cover of G'. Therefore the vertex cover size of G’ is bounded by 3|T'| + 4. See
Figure for an illustration. We now claim that (G, k) is a yes-instance of RBDS if and
only if (G', k') is a yes-instance of DEFENSIVE ALLIANCE.

Suppose there exists a vertex set X C .S of size at most k& in G such that every vertex in T’
has at least one neighbour in X. We claim that the set R = S;U {30 €S |se X} UToU{z*}
is a defensive alliance in graph G’. Let = be an arbitrary element of R. We prove that x is

protected in R.

Case 1: Suppose x € S;. Note that Ngr(z) = Np,(x) U {z*}. Thus, including itself, it has
dg(z) 4+ 2 defenders in G'. The attackers of = consist of elements of Np () and element
a. Hence z has dg(x) 4+ 1 attackers. This shows that x has at least as many defenders as

attackers; hence x is protected.

Case 2: Suppose z € {so € Sy | s € X }. Note that Ng(z) = N, (x). Thus, including itself,
it has dg(z) 4+ 1 defenders in G'. The attackers of = consist of elements of Nr,(z). Hence «

has dg(z) attackers in G’. This shows that x is protected.
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Figure 4.2: PPT from RBDS to DEFENSIVE ALLIANCE, where graph G is show on the left
and G’ is shown on the right.

Case 3: Suppose © € Ty. Clearly, including itself, x has 2dg(z) + 3 neighbours in G'.
Thus it requires at least dg(z) + 2 many defenders in G’. Note that, including itself, = has
dg(x) + 1 neighbours in Sy C R. Therefore, it requires at least one neighbour from the set
{so €S |seX } inside the solution and this is true because (G, k) is a yes-instance of
RBDS.

Case 4: Suppose x = x*. It has the same number of defenders and attackers in G’. This

shows that x is protected.

Conversely, suppose there exists a defensive alliance R of size at most k&’ in G’. Clearly,

no vertex from the set Q = 73 UTy U {a,b,c} |J V; can be part of R as |R| < k' and
teT1UTs

de(v) > 2k for all v € Q). Since R is non-empty, it must contain a vertex from one of the
sets {z*}, 51, T or Sp.
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Case 1: Suppose z* € R. Since x* has |S| many neighbours in @, it implies that all the
neighbours of z* in S; must be inside the solution for protection of x*. This implies that
S1 € R. Let v be an arbitrary vertex in S;. Note that v has dg(v) neighbours in Tj, and
it has dg(v) + 1 neighbours in ). For protection of v all the neighbours of v in Ty must be
part of the solution. This implies that Ty C R as all the vertices in S| must be protected.
Note that till now we have added |S| + |T| + 1 many vertices in the solution. Therefore,
we can add at most k vertices to the solution from the set Sy as otherwise the solution size
will exceed k’. Suppose we add a set X C Sy of size at most k to the solution. Consider
the protection of vertices in Ty. If v is a vertex of T, then it has dg(v) neighbours in S
and similarly dg(v) neighbours in S;. Excluding itself, v has 2dg(v) 4+ 2 neighbours in G'.
Thus it requires at least dg(v)+ 1 many neighbours inside the solution. We know that dg(v)
neighbours are inside the solution due to the fact that S; C R. Therefore, it requires at least
one neighbour from Sy inside the solution. Since there exists a set X C S of size at most k
such that all the vertices in Ty are protected, it shows that all vertices in T have at least

one neighbour in X.

Case 2: Suppose R contains a vertex v from the set S7. In this case, the protection of v
requires x* to be inside the solution and then the same argument as in Case 1 will lead to

the proof.

Case 3: Suppose R contains a vertex v from the set Ty. Excluding itself, v has 2dg(v) + 2
neighbours in G’. Thus it requires at least dg(v) + 1 many neighbours from Sy U S; inside
the solution. This implies that at least one neighbour from the set S; must be inside the

solution. Now the same argument as in Case 2 will lead to the proof.

Case 4: Suppose R contains a vertex v from the set Sy. Clearly, it has dg(v) neighbours
in 7o C @ and dg(v) neighbours in 7j. Since the vertices in the set ) cannot be part of
the solution, the protection of v will imply that all the neighbours of v in Tj are part of the
solution. In other words, there exists a vertex in Ty which is inside the solution. Now the

same argument as in Case 3 will lead to the proof.

This proves that (G, k) is a yes-instance of RBDS. O
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4.3 DEFENSIVE ALLIANCE has no Subexponential Algo-

rithm

In this section, we prove lower bound based on ETH for the time needed to solve the DE-
FENSIVE ALLIANCE problem. In order to prove that a too fast algorithm for DEFENSIVE
ALLIANCE contradicts ETH, we give a reduction from VERTEX COVER in graphs of maxi-
mum degree 3 and argue that a too fast algorithm for DEFENSIVE ALLIANCE would solve
VERTEX COVER in graphs of maximum degree 3 in time 2°0®. Using Theorem 1.1 in 185],
we get that there is an algorithm which finds a minimum size vertex cover in an arbitrary
graph on n vertices in time sub-exponential in n if and only if there is an algorithm which
finds a minimum size vertex cover for graphs that have maximum degree at most 3 in time
sub-exponential in n. Assuming ETH, there is no algorithm which finds a minimum size
vertex cover in an arbitrary graph on n vertices in time sub-exponential in n [100]. This
implies, assuming ETH, there is no algorithm which finds a minimum size vertex cover for

graphs that have maximum degree at most 3 in time sub-exponential in n.

Theorem 4.3.1. Unless ETH fails, DEFENSIVE ALLIANCE does not admit a 2°™ algorithm

where n is the number of vertices of the input graph.

Proof. We give a linear reduction from VERTEX COVER in graphs of maximum degree 3 to
DEFENSIVE ALLIANCE, that is, a polynomial-time algorithm that takes an instance (G, k)
of VERTEX COVER, where G has n vertices and m = O(n) edges, and outputs an equivalent
instance of DEFENSIVE ALLIANCE whose size is bounded by O(n). We assume that the input
graph contains at least two edges otherwise the problem will be polynomial time solvable.
We construct an equivalent instance (G’, k') of DEFENSIVE ALLIANCE the following way.
See Figure [4.3]for an illustration.

1. We introduce the vertex sets X and Y into G’, where X = V(G) = {vy,...,v,} and
Y = E(G) = {e1,e,...,en}, the edge set of G. We make v; adjacent to e; if and only

if v; is an endpoint of e;.
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Figure 4.3: The reduction from VERTEX COVER to DEFENSIVE ALLIANCE.

2. For every 1 < ¢ < m, we introduce a cycle C; of length 4. For every 1 <1 < m — 1,
make every vertex of C; adjacent to e; and e;;1; and make every vertex of C,, adjacent

to e,, and e;.

3. We add a set F' = {fi, f2,..., fs} of 8 new vertices into G'. Set k¥’ = 5m + k. For
every vertex f € F' we introduce a set V; of 4k’ new vertices into G’ and make them
adjacent to f. We make every vertex of {fi, fa, fs, f1, f5} adjacent to every vertex of

C; fori=1,2,...,m. We also make every vertex of I’ adjacent to every vertex of Y.

4. Finally, we introduce a vertex a and make it adjacent to every vertex of X U |J V.
fEF

We now argue equivalence of the instances. Suppose there exists a vertex cover S of size

at most k in G. We show that D = SUY |J V(C;) is a defensive alliance of size at most &’
i=1
in G'. It is easy to verify that all the vertices in D are protected.

To prove the reverse direction of the equivalence, suppose now that D is a defensive

alliance of size at most &’ in G’. Clearly, no vertex from @ = {a} U F U (J V; can be part
fer
of D. To prove the reverse direction, we need the following simple claim:

Claim 4.3.1. Every defensive alliance D of G' contains the set'Y |J V(C;).
i=1
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Proof. Since the defensive alliance is non-empty, it must contain a vertex from XUY G V(C;).
i=1

Case 1: Suppose D contains e; from Y. We observe that deg.,(e;) = 18. Note that eight

neighbours of e; in F' cannot be part of the solution as they belong to the forbidden set

Q. This implies that we need to add at least one vertex from the set V(C;_;) U V(C;) to

the solution for the protection of e;. Without loss of generality, suppose we include one

vertex from V(C;) in the solution. Inclusion of one vertex from the set V(C;) in the solution

forces V(C;) € D. This in turn forces e;;; in the solution. Repeatedly applying the above
argument, we see that Y |J V(C;) C D.
i=1

Case 2: Suppose D contains an arbitrary vertex from the set X (J V(C;). Then the pro-
i=1
tection of that vertex forces at least one vertex from Y in the solution. Using the argu-

ment in Case 1, it implies that Y |J V(C;) € D. This completes the proof of the claim.
i=1

J

We observe that for every vertex e € Y, we have included eight out of its 18 neighbours in
the solution. For the protection of e, we need to include at least one more of its neighbours
from X in the solution. As we have already added 5m vertices in the solution, we can add a
set S C X of at most k vertices in D such that every vertex e € Y has at least one neighbour
in S. If such a set S exists then it forms a vertex cover of G. This shows that (G, k) is a
yes-instance of VERTEX COVER. n

4.4 Defensive Alliance on Circle Graphs

Recall that a circle graph is the intersection graph of a set of chords of a circle. That is,
it is an undirected graph whose vertices can be associated with chords of a circle such that
two vertices are adjacent if and only if the corresponding chords cross each other. Here, we
prove that DEFENSIVE ALLIANCE is NP-complete even when restricted to circle graphs, via
a reduction from DOMINATING SET. It is known that DOMINATING SET on circle graphs is
NP-hard [88].

Theorem 4.4.1. DEFENSIVE ALLIANCE on circle graphs is NP-complete.
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On the way towards this result, we provide a hardness result for a variant of DEFEN-
SIVE ALLIANCE which we require in the proof of Theorem The problem DEFENSIVE

ALLIANCEF

generalizes DEFENSIVE ALLIANCE where some vertices are forced to be outside
the solution; these vertices are called forbidden vertices. This variant can be formalized as

follows:

DEFENSIVE ALLIANCEF

Input: An undirected graph G = (V| E), a positive integer r and a set V7 C V(G) of
forbidden vertices.
Question: Is there a defensive alliance S C V such that 1 < |S| <r, and SNV = (?

Lemma 4.4.2. DEFENSIVE ALLIANCEF

on circle graphs is NP-complete.

Proof. Tt is easy to see that the problem is in NP. To show that the problem is NP-hard
we give a polynomial reduction from DOMINATING SET on circle graphs. Let (G, k) be an
instance of DOMINATING SET, where G is a circle graph. Suppose we are also given the
circle representation C' of G. Without loss of generality, we can assume that none of the
endpoints of chords overlap with each other. We create a graph G’ and output the instance
(G', Vo, K'). See Figure The steps given below describe the construction of G':

e Step 1: Take two distinct copies G; and Go of G and let v; be the copy of v € V(G)
in graph G;. For each v € V', make v; adjacent to every vertex of Ng,[vs] and similarly
make vy adjacent to every vertex of Ng, [vi]. Note that this operation can be easily
incorporated in the circle representation by replacing the chord corresponds to v with

two crossing cords correspond to v; and vy as shown in Figure

e Step 2: For every v € V, create two sets of vertices X = {af,...,25,,,} and
YU ={y},...,y5,.1} and make vy, v, adjacent to every vertex of X¥ UY". This can
be easily incorporated in circle representation by introducing 2n + 1 parallel chords for
the vertices x7,...,x5,,, which cross the chords for vy, vy. Similarly, introduce 2n + 1

parallel chords for the vertices 47, ..., 5, which cross the chords for v, v, as shown
in Figure

e Step 3: For each zv € XV, create two 3-vertex cliques C!, and C?,, and make zV

adjacent to every vertex of C}, and C2,. For 1 < i < 2n, make every vertex of C},

87



aq a9

Z)] [)2

Figure 4.4: (i) Graph G and its circle representation. (ii) The graph produced after the
first step of reduction and its circle representation.

Figure 4.5: Hlustration of Step 2. Here 2n +1 = 7.

adjacent to every vertex of C;;,H. Similarly, make every vertex of C’%y adjacent to every
vertex of ngﬂ for 1 < i < 2n. For each y* € Y, create two 3-vertex cliques C;v and
Cgv, and make y¥ adjacent to every vertex of C’;v and Cgv. Make every vertex of C’Z};_J
adjacent to every vertex of C’lh1 for 1 < i < 2n. Similarly, make every vertex of 02;,
adjacent to every vertex of C;E,H for 1 < i < 2n. We start at an arbitrary vertex on
the circle representation of C' of G and then traverse the circle in counter clockwise
direction. We record the sequence in which the chords are visited. For example, in
Figure i), if we start at the red vertex on the circle, then the sequence in which the
chords are visited, is a, ¢, b, a, ¢, b. Note that every vertex appears twice in the sequence
as every chord is visited twice while traversing the circle. Thus we get a sequence S
of length 2n where n is the number of chords. We use the sequence to connect newly
added cliques. For every consecutive pair (u,v) in the sequence S, make every vertex
of ngn+1 adjacent to every vertex of C;SHH when both v and v appear for the first time
in the sequence S; make every vertex of ngnH adjacent to every vertex of C 1571“ when
both u and v appear for the second time; and make every vertex of ngnﬂ adjacent to

every vertex of C’ylg » when v appears for the first time and v appear for the second

88



- a
C? Yon+1

a y{2'11,+1
" Yan+1 9
Yont1 ) * P Yont1 b
Yon+1
! 1
Y, )
e Vy'l_)ln+l
lb ® A C’217
Yon+1 yb Yan+1
2n+1

2
b
Yon+1

Figure 4.6: Illustration of Step 3.

time. These adjacencies are shown in green color in Figure and

e Step 4: For every vertex u in cliques, add d forbidden vertices where d is the degree of
u until now in G’ and make them adjacent to u. For every vertex u € X" UY", add six
forbidden vertices and make them adjacent with u. For very vertex u € V(G1)UV (Gs),
add 4n + 3 forbidden vertices and make them adjacent to w. This completes the
construction of G'. We set k' = Tn(4n +2) +n+ k and Vi be the set of all one degree

forbidden vertices.

We observed that the constructed graph G’ is indeed a circle graph, and the construction
can be performed in time polynomial in n. We now claim that G admits a dominating set
of size at most k if and only if G’ admits a defensive alliance D of size at most &’ such that
DN Va=(. Assume first that G admits a dominating set S of size at most k. Let

2n+1
c= UJ UYveuve)uvic,) uv(c).
veV(G) i=1

Consider D = CU {vl Lve S} UV(G)U U X*UY?. Clearly, |D| < Tn(4n+2) +n+k
veV(Q)
and D NV = (), so it suffices to prove that D is a defensive alliance in G'. We observe

that every vertex in C has equally many neighbours inside and outside the solution. Every

vertex v € |J XVUY" is protected as it has at least 7 neighbours inside the solution and
veV(Q)

at most 7 neighbours outside the solution. Each v € {Ul v E S} U V(Gs) has at least
d+ 1+ 4n + 2 neighbours inside the solution and at most d + 1 + 4n + 2 neighbours outside
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G/

Figure 4.7: The reduction of an instance G of DOMINATING SET on circle graphs to an

instance G’ of DEFENSIVE ALLIANCE! in Theorem |4.4.1] Here 2n +1 = 7. One degree
forbidden vertices introduced in Step 4 are not shown here.
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the solution where d = dg(x). This shows that D is a defensive alliance of size at most &’ in

G

Conversely, suppose that G’ admits a defensive alliance D of size at most k' such that
DNV =0. We define
Va=Cu |J Xxvuvy
veV(Q)
We first show that VA C D. Since D in non-empty, it should contain a vertex from either
Va or V(G1) UV (Gy). We consider the following cases:

Case 1: Suppose D contains a vertex from C. Without loss of generality, we may assume
that D contains a vertex u from V(CL,). It is easy to see that u is protected if and only if
all its non-forbidden neighbours are inside D because the number of forbidden neighbours
of u is equal to the number of non-forbidden neighbours. This implies that ¥ € D. It
is easy to note that either C,o € D or Cpo N D = (). Since dg/(2¥) = 15 and z¥ has 6

forbidden neighbours, the above observation implies that C, U C?, C D. This implies that
2n+1

Cl,UC?% C D. This in turn implies that X" C D. Note that every vertex of Cig .
Z:l 3 K3 n
is adjacent to every vertex of C;QM " (resp. C’yléu +1) for some w € G such that u,w are con-

1

secutive elements in the sequence S and w appears for the first (resp. second) time in the

sequence. Therefore 261 C;;u U C’i? C D and also X* C D if w appears for the first time
= 2n+1

in the sequence; whereas | J C'ylgﬂ U 052” C D and also Y* C D if w appears for the second

time in the sequence. Reple:altedly applying the above argument, we get Vo C D.

Case 2: Suppose D contains a vertex from [ J X“UY". Without loss of generality, we may
veV(Q)
assume that D contains z¥ from X". We observe that the protection of z¥ clearly requires

at least one vertex from the set Cl, U CZ,. Now, Case 1 implies that Vo C D.

Case 3: Suppose D contains a vertex v from V(G;) U V(Gy). The protection of v requires
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Figure 4.8: The circle representation to get rid of forbidden vertices when k' = 2.

at least one vertex from the set X" U Y". Now, Case 2 implies that Vo C D.

Observe that |[Va| = Tn(4n + 2). Therefore |D N (V(Gy) U V(G2))| < n+ k. For each
v € V(G), the protection of every vertex in X¥ U Y" requires either v; or vy inside the
solution. Since, v; and vy are twins, we can assume that V(Gy) C D. Let vy € V(Gy). We
see that vy has d + 1 + 4n + 2 neighbours (including itself) inside the solution. The vertex
v9 has 4n + 3 forbidden neighbours. The only unsettled neighbours of vy are in V(G) and
vy has d + 1 neighbours in V(G;). For protection of each vy € V(Gy), we require at least
one neighbour from V(G;) inside the solution. We can add at most k vertices from V(Gy)
to the solution as we have already added 7n(4n + 2) + n vertices. Clearly, S = V(G1) N D

is a dominating set of size at most k. O

4.4.1 Proof of Theorem [4.4.1

It is easy to see that the problem is in NP. To show that the problem is NP-hard we give
a polynomial reduction from DEFENSIVE ALLIANCEY . Let (G,k, V) be an instance of

DEFENSIVE ALLIANCEF

, where G is a circle graph. We construct an instance (G', k') of
DEFENSIVE ALLIANCE the following way. For every x € V4, create a vertex 2’ and a set
of 2k’ vertices V5. Make both x and z’ adjacent to every vertex in V. This completes the

construction of G'. Set k' = k.

We observe in Figure that the constructed graph G’ is indeed a circle graph, and
the construction can be performed in time polynomial in n. We now claim that G admits
a defensive alliance D of size at most k such that D N V5 = @ if and only if G’ admits a
defensive alliance D’ of size at most k’. Assume first that D is a defensive alliance of size at
most &k in G such that D N'Vy = (). Consider D’ = D. Clearly, D’ is a defensive alliance of
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size at most k' in G'. Conversely, suppose that G’ admits a defensive alliance D’ of size at
most k'. Observe that D'N |J VEU{x,2'} = 0. As x and 2’ are of degree 2k, they cannot

zeVg
be part of a defensive alliance of size at most &’. As x and 2’ are outside D', the vertices in

V& cannot be in D'. Consider D = D'. Clearly, D is a defensive alliance of size at most k in
G such that DN V5 = 0. |

4.5 Closing Remarks and Future Directions

In this work we proved that the DEFENSIVE ALLIANCE problem is W|1]-hard parameterized
by a wide range of fairly restrictive structural parameters such as the feedback vertex set
number, pathwidth, treewidth, treedepth, and clique width of the input graph, even when
restricted to bipartite graph. We also proved that the problem parameterized by the vertex
cover number of the input graph does not admit a polynomial compression unless coNP C
NP /poly; it cannot be solved in time 2°("), unless ETH fails, and the DEFENSIVE ALLIANCE
problem on circle graphs is NP-complete. By the construction of our proofs in Section|4.1] it
is clear that hardness also holds for problem variants that ask for defensive alliances exactly
of a given size. In the future it may be interesting to study if our ideas can be useful for
different kinds of alliances from the literature such as offensive and powerful alliances. The
parameterized complexity of defensive alliance problems remain unsettled when parameter-
ized by other important structural graph parameters like twin cover and modular-width. It
will also be interesting to study W|¢]-membership of DEFENSIVE ALLIANCE problem param-
eterized by treewidth. In [18]|, Hans L. Bodlaende, Gunther Cornelissen and Marieke van der
Wegen put up an open question that whether DEFENSIVE ALLIANCE is XNLP-hard when
parameterized by treewidth. Note that this would imply that DEFENSIVE ALLIANCE is
W|t]-hard for all t when parameterized by treewidth of the input graph. It is also interesting
to see if DEFENSIVE ALLIANCE is W[1|-hard when parameterized by stable gonality [18].
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Chapter 5

Locally Minimal Defensive Alliance in

Graphs

This chapter deals with the problem LOCALLY MINIMAL DEFENSIVE ALLIANCE: Given an
undirected graph G = (V, F) and an integer k, the question is whether there is a vertex subset
D C V of at least k vertices satisfying the following two conditions: 1. Each x € D has at
least as many neighbors (including itself) in S as it has neighbors not in D. 2. Removing any
vertex from D destroys Condition 1 for at least one remaining vertex in D. A vertex subset
of any size fulfilling the first condition is called a defensive alliance. Clearly, the vertex set of
the whole graph is a defensive alliance. Any defensive alliance satisfying the second condition
is called a locally minimal defensive alliance. LOCALLY MINIMAL DEFENSIVE ALLIANCE
is known to be NP-hard and its parameterized complexity has been studied before, but the
question whether it is FPT when parameterized by the solution size k has still remained
open. This question is answered positively here. Further results are a kernel with k9%
vertices on Cs-free and C;-free graphs of minimum degree at least two and a subexponential
algorithm with respect to k for planar graphs of minimum degree at least 2. This chapter is

based on the paper [69].

5.1 Main Results and Proof Techniques

Theorem 5.1.1. LOCALLY MINIMAL DEFENSIVE ALLIANCE s FPT.
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The proof of Theorem relies on a win/win argument. We first find the diameter of
G in polynomial time. We show that if diam(G) > 4k? then the given instance (G, k) is
a yes-instance (see Theorem . Hence, we assume that diam(G) < 4k* If G has a
high-degree vertex v such that v has a large number of neighbours of degree > 2, then we
show that G has a locally minimal defensive alliance of size at least k£ (Lemma . If
G has a high-degree vertex v such that v has a small number of neighbours of degree > 2,
then we give an FPT algorithm to check whether there is a locally minimal defensive alliance
containing v (Lemma . If G has no high-degree vertices, then as diam(G) < 4k?, the
number of vertices in GG is bounded by a function of £ alone, and we are done. Our algorithm
starts with trivial defensive alliance V' (G) and tries to construct a locally minimal defensive
alliance of size at least k in a greedy fashion. Either we succeed in this process or derive some
useful structural properties of the greedy solution. The difficult part is to keep witnesses of

minimality for our solution. This is highly non-trivial and uses several interesting ideas.

The running time of our algorithm on general graphs is quite large and thus we also study

the problem on special graph classes and obtain exponential kernels in polynomial time.

Theorem 5.1.2. LocALLY MINIMAL DEFENSIVE ALLIANCE on Cs-free or Cy-free graphs

of minimum degree at least 2, admits a kernel with at most k®%) vertices.

The proof of the above theorem uses some subtle structural observations on these special
graph classes which allows us to bound the maximum degree of the input graph by O(k?)
to obtain the kernel mentioned in Theorem [5.1.2] The next special class of graphs that we

consider are planar graphs. On this class we obtain a subexponential time algorithm.

Theorem 5.1.3. LOCALLY MINIMAL DEFENSIVE ALLIANCE on the planar graphs of min-

imum degree at least 2, admits an FPT algorithm with running time KOWR),

The proof of this theorem relies on a win/win argument that exploits the relation between
treewidth and the linear grid theorem [54]. We show that the existence of “I';, .,” as
a minor obtained by only edge contractions guarantees the existence of locally minimal
defensive alliance of size at least k. When this case does not arise, we use a dynamic
programming algorithm on graphs of treewidth at most O(v/k) and maximum degree A
with running time A°VHROW  Finally, to get the required time complexity mentioned in
Theorem we show that if the maximum degree of the input graph is at least O(k?),

then there always exists a locally minimal defensive alliance of size at least k.
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Finally, getting rid of the minimum degree 2 requirement in both Theorems and
are interesting open questions.

5.2 FPT algorithm parameterized by solution size

Let D be a defensive alliance in G. A vertex u € D is said to be a good vertex if it has at
least one marginally protected neighbor in D. A vertex u € D is said to be a bad vertex if
it has no marginally protected neighbor in D. In this section we propose a simple greedy
algorithm (Algorithm that takes as input a defensive alliance and returns a locally minimal
defensive alliance. At each iteration of Algorithm[I} if D contains a bad vertex, we identify
it and remove from D. During the execution of Algorithm (I} a good vertex may become a

bad vertex and finally gets removed from D. For an illustration, see the graph in Figure

@) mp (V) bad
D)
) mp 1) bad

Figure 5.1: Left hand side figure shows a graph GG and a defensive alliance D. The vertices
of D are shown in green; u is a bad vertex where as v and w are good vertices. Right hand
side figure shows the defensive alliance after the first iteration of Algorithm |1} Note that
initially v and w are good vertices but after the first iteration of Algorithm [1} they become
bad vertices.

The vertices of defensive alliance D are shown in green. Here u is marginally protected
(mp) where as v and w are over protected (op); v and w are good vertices as they have a
marginally protected neighbour u; on the other hand w is a bad vertex as it has no marginally
protected neighbour in D. At the end of the first iteration, u is removed from D as u is a bad
vertex. Then v and w become bad vertices as they have no marginally protected neighbour
in the updated D. Finally, v and w are also removed from D. We now introduce the notion

of crucial vertices in a defensive alliance.

A good vertex is called a crucial vertex of D if it is not removed from D during the exe-
cution of Algorithm [I] Consider the graph in Figure[5.2] During the execution of Algorithm
only uy will be removed from D; uq, us, ug will not be removed from D. Thus uy, us, us

are crucial vertices. Note that u; and uy are adjacent and marginally protected in D.
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Figure 5.2: A graph G and a defensive alliance D = {uy, us, u3, us}. Here uy, us and us are
crucial vertices of D.

Now we characterize crucial vertices in a defensive alliance D. Suppose u; and us are two
marginally protected and adjacent vertices in D. Let Np({u1,us2}) be the set of neighbours
of {uy,us} in D. It is easy to note that during the execution of Algorithm (1| the vertices of
{uy,us} U Np({us, us}) remain good and hence never removed from D. Thus the vertices in

{uy,us} U Np({uy,us}) are crucial vertices. More formally, we have the following definition:

Definition 5.2.1. Let D be a defensive alliance in G. A vertex u € D is said to be a crucial
vertex of D if u has a marginally protected neighbor u; in D and wu; also has a marginally

protected neighbor us in D (up and u are not necessarily distinct vertices).

Lemma 5.2.1. A defensive alliance D of size at least two is a locally minimal defensive

alliance in G if and only if every vertex of D is crucial.

Proof. Suppose D is a locally minimal defensive alliance of size at least two in G. For
any u € D, D\ {u} is not a defensive alliance. This implies that « must have a marginally
protected neighbor u; in D. Due to the same reason u; must also have a marginally protected
neighbor uy in D. Therefore u is a crucial vertex in D. On the other hand, suppose every
vertex of D is crucial. Therefore, for any u € D, D\ {u} cannot form a defensive alliance as

u is adjacent to a marginally protected vertex in D. ] We

now propose a simple greedy algorithm that takes as input a defensive alliance and returns

a locally minimal defensive alliance.

During the execution of Algorithm |1, a good vertex may become a bad vertex and finally
gets removed from D. Similarly during the execution of Algorithm |1} a good or bad vertex

may become a crucial vertex, and then it never gets deleted from D. For example, see Figure

B3l

Lemma 5.2.2. Let u be a crucial vertex in D;,. Then the locally minimal defensive alliance
D obtained by Algom'thm contains u.
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Algorithm 1
Require: A graph G = (V, F) and a defensive alliance D;,, in G
Ensure: A locally minimal defensive alliance D
. D =D,
while D contains a bad vertex do
Identify a bad vertex u € D
D =D\ {u}
end while
return D

Proof. As u is a crucial vertex in D;,, u has a marginally protected neighbor u; in D;, and
up also has a marginally protected neighbor us in D;,. As u; has a marginally protected
neighbor uy in D;, and similarly us has a marginally protected neighbor w; in Dy,, uy,us
will never be removed during the execution of Algorithm|l| As u has a marginally protected
neighbor u; which will never be removed, u will also never be removed from D;,. Thus the
locally minimal defensive alliance D obtained by Algorithmcontains all crucial vertices of
D;,. O

We now modify Algorithm Note that in Algorithm [Il the bad vertices of D were
getting removed from D, in an arbitrary order. In the following algorithm, we restrict the
order in which the bad vertices will be removed from D;,. First, the bad vertices of D, \ C
will be removed, and then the bad vertices of C' will be removed. Note that due to Lemma
we know that if there exists a defensive alliance D;,, that contains k crucial vertices
then we have a locally minimal defensive alliance D of size at least k. The importance of

introducing the set C' is explained in the next lemma.

Algorithm 2
Require: A graph G = (V. E), a defensive alliance D C V(G) and a set C' C D.
Ensure: A locally minimal defensive alliance.
while D \ C contains a bad vertex of D do
Identify a bad vertex u € D\ C
D= D\ {u}
end while
run Algorithm|1jon (G, D).

Lemma 5.2.3. Suppose Dy is the defensive alliance obtained at the end of the while loop
in Algorithm @ Then every vertex in Do \ N[C] is a crucial vertex of Dy. Furthermore,

every vertex in Do \ N[C] will be there in the locally minimal defensive alliance obtained at
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(¢) Initially D = Dy, (i7) Tter. 1: D = {wvy,v9,v3,v4} (i4i) Tter. 2: D = {vy, v9,v3}

Figure 5.3: The execution of Algorithmon the input graph G and D;,, = {vg, v1, v, U3, v4}.
In each iteration, green vertices are in D and red vertices are outside D. (i) Initially the
vertices vy, vy, v3, vy are good in Dy, as they have a marginally protected neighbor vy. On the
other hand, vy is a bad vertex in Dj, as it has no marginally protected neighbor in D;,, (note
that vertices vy, v9, v3, v4 are overprotected in Dy,). (ii) In the first iteration, we remove the
bad vertex vy and get D = {vy,vq, v3,v4}. All the vertices of D are bad. (iii) In the second
iteration, we remove say vy and get D = {vy,v9,v3}. Note that all the vertices of D are
good as well as crucial in D now. Thus the algorithm stops and returns D = {vy, vo,v3}, a
locally minimal defensive alliance. It is important to note that initially, v, was a good vertex
in D = D;,, but it has been removed from D during the execution of the algorithm. Thus
during the execution of Algorithm[I] a good vertex may become bad and get deleted; but a
crucial vertex will never be deleted.

the end of Algorithm

Proof. Let u be an arbitrary vertex in Do\ N[C]. Since every vertex in Dy\C has a marginally
protected neighbor in Dy, u also has a marginally protected neighbor w; in Dy. Clearly, u;
is in Dy \ C. Therefore u; also has a marginally protected neighbor us (us can be u) in Dj.
By Definition u is a crucial vertex in Dy. Therefore every vertex in Dy \ N[C] is a
crucial vertex. Due to Lemma [5.2.2| since every vertex in the set Dy \ N[C] is crucial in Dy,
applying Algorithm [T will return a locally minimal defensive alliance which contains the set
Dy \ N[C]. O

Next, we show that a correct choice of C' in Algorithm (2| can generate a locally minimal

defensive alliance of size at least k in G.
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5.2.1 Graphs with diameter > 4k?

Let G be a graph with diam(G) > 4k*. In this section, we prove that G contains a locally

minimal defensive alliance of size at least k. In particular, we prove the following theorem.

Theorem 5.2.4. Let k be a positive integer and S;, be a defensive alliance in G. If
diam(G[S;,]) > 4k%, then G has a locally minimal defensive alliance of size at least k.

Moreover, such a locally minimal defensive alliance can be obtained in polynomial time.

We will begin by applying Algorithm |3 on defensive alliance Sj,.

Algorithm 3
Require: A graph G = (V, E) and a defensive alliance S;,, in G
Ensure: A modified defensive alliance S
1: S=25;,
2: while S has either a degree one bad vertex or a bad vertex with a degree one neighbor
in S do

3: for each u € S do

4: if u is a bad vertex and deg,(u) = 1 then
5: S =5\ {u}

6: end if

7: end for

8: for each v € S do

9: if v is a bad vertex and v has a neighbor say w in S with deg.(u) = 1 then
10: S =5\{v}

11: end if

12: end for

13: end while

14: return S

Lemma 5.2.5. Let S be the defensive alliance obtained at the end of Algorithm[3 Let
u,v € S be two adjacent vertices such that deg,(u) = 1 and v be the only neighbor of u in

G. Then both u and v are crucial.

Proof. As u is a degree one vertex, its only neighbor v € S must be marginally protected;
otherwise, u is a bad vertex and the for loop of line 2-6 in Algorithm [3]would have deleted
u. Note that u is overprotected. Thus v must be adjacent to a marginally protected vertex
w in S; otherwise the for loop of line 7-11 in Algorithm Would have deleted v. This shows

that both u and v must be crucial in S. This completes the proof of the lemma. m
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Let S be the defensive alliance obtained at the end of Algorithm (3| Note that G[S] may

be disconnected. Suppose S, Ss, ..., are the connected defensive alliances such that S =
!

U Si.

i=1

Lemma 5.2.6. For eachi € [l], Algom'thm on (G, S;) produces a locally minimal defensive

alliance of size of at least two.

Proof. For the sake of contradiction, suppose Algorithm [1{ on (G, S;) produces a locally
minimal defensive alliance {u} of size exactly one. It is easy to see that u must be a degree
one vertex. Suppose the only neighbor of u is v. By Lemma|[5.2.5] both u and v are crucial
vertices in S;. So the connected locally minimal defensive alliance obtained by Algorithm
on (G, S;) contains both u and v, a contradiction to the assumption that it is of size exactly

one. This proves the lemma. O

Lemma 5.2.7. Suppose S;, is the input defensive alliance in Algorithm @ Suppose the
diameter of graph G|[Si) > 4k?, that is, there exists a pair of vertices u,v € Sy, such that
d(u,v) = 4k* in G[Sin). Let P = (u = vy, v1,09, ..., 042 = v) be a shortest u — v path in
G[Sin]. Then for each 1 < i < 4k? — 1, either v; € S or v;y1 € S where S is the defensive
alliance obtained at the end of Algorithm 3}

Proof. For the sake of contradiction suppose that both v; and v;;; are not in S for some
1 <i < 4k*—1. As both v; and v;,; have a degree of at least two, they must have been
deleted by Algorithm [3]during the execution of second for loop of lines 8-12. Observe that
v; and v;;1 must have at least one neighbor of degree one. As these degree one neighbors
are not deleted during the execution of the first for loop, it implies that v; and v, y; are
marginally protected. As v; is adjacent to v; 1, it is clear that both v; and v;,; are crucial.
Therefore Algorithm cannot delete either of them. This is a contradiction. O]

Due to Lemma m, it is clear that if diam(G[S;,]) > 4k? either S contains a connected
defensive alliance S; of diameter at least 8% or a collection of at least g connected defensive
alliances S1,S5,...,5;. In the latter case, it is easy to see that one can obtain a locally
minimal defensive alliance of size at least two by simply applying Algorithm on (G, S;) for

ecach 7. This is true due to Lemma [5.2.6

Lemma 5.2.8. If there is a connected defensive alliance S; of diameter at least 8k then there

exists a locally minimal defensive alliance of size at least k.
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Proof. As diam(G[S;]) > 8k, there exists a pair of vertices wy and wgy such that d(wg, wgy) =
8k in G[S;]. Let P = (wq, w1, . .., wsk) be a shortest wg-wsy, path in G[S;]. Set C' = {wy; | 0 <
i < 2k}. Run Algorithm [2| on (G, S;,C). Let S;(C) be the defensive alliance obtained at
the end of the while loop of lines 1-4 in Algorithm Note that S;(C') may not be a
connected defensive alliance. Suppose G[5;(C1)], G[Si(C2)], ..., G[Si(Cy)] are the connected
components of S;(C') which generates a partition of C' into p parts Cy,Cy, ..., C, such that
C; C S;(C;) for all j € [p]. Line 5 of Algorithmexecutes Algorithmon (G, S;(C)), that
is, it executes Algorithm[LJon (G, S;(C;)) for all j € [p].

Claim 5.2.1. Line 5 of Algom'thm@ runs Algom'thm on (G, S;(C))) and outputs a locally
minimal defensive alliance S;; such that |S;;| > max {2, |C;| — 1} for all j € [p).

Proof. Due to Lemma we always have [S;;| > 2 as S;(C;) C S;. We now prove that
Algorithm [1| on input instance (G, S;(C;)) returns a locally minimal defensive alliance S;;
of size at least |C;| — 1. Suppose C; = {c1,...,¢}, ¢ > 2. We know d(c;, ¢i41) > 4 and
d(wo, ¢1) < d(wo, ci41) in G[S;(Cj)] forall 1 <1 < g—1. As G[S;(C};)] is connected, a shortest
path between ¢; and ¢4 in G[S;(C})] contains a vertex, say x, such that = € S;(C;) \ N[C}].
By Lemma z is a crucial vertex in S;(C;). As C; has ¢ vertices, we have at least
q — 1 crucial vertices in S;(C;). By Lemma[5.2.2] the locally minimal defensive alliance S;;
returned by line 4 of Algorithm |2| contains all ¢ — 1 crucial vertices of S;;. Thus we have
|Si;| > ¢ — 1. This completes the proof of the claim.

P

As the closed neighborhood of one S;; does not intersect others and |C| > 2k, we get |J S;
j=1

is a locally minimal defensive alliance of size at least k. This completes the proof of the

lemma. O

5.2.1.1 Proof of Theorem|5.2.4

Proof. We present a polynomial-time algorithm to obtain a locally minimal defensive alliance
l

of size at least k. We begin by applying Algorithm [3[on (G, S;,). Let S = |J S; be the
i=1

defensive alliance obtained at the end of Algorithm [3| where Si,55,...,S; are connected

defensive alliances such that N[S;] N S; = 0 for all 4,5 € [I] and ¢ # j. Due to Lemma
we know that applying Algorithm [1f on each S; returns a locally minimal defensive
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alliance of size at least 2. Therefore we can assume that [ < g, otherwise it is a yes instance.
As diameter of G[S] > 4k? and | < £, due to Lemma [5.2.7| we observe that there exists
i € [l] such that diam(G[S;]) > 8k. Due to Lemma@ one can obtain a locally minimal
defensive alliance of size at least k& by applying Algorithm on (G, S;). As Algorithm
and [3] run in polynomial time, the above algorithm runs in polynomial time as well. This

completes the proof of Theorem [5.2.4] O

5.2.2 Graphs with diameter < 4k?

Let G be a graph with diam(G) < 4k?. We consider two cases: A(G) is large and A(G) is

small.

5.2.2.1 A(G) is large:

We partition the set of vertices of G into two parts H and L. We define
H ={veV(G) | deg(v) > 2fo(k)} and L ={veV(G)|deg(v) <2fo(k)}

where fo(k) = k*°" for some large constant c¢g. The vertices of H are called high-degree
vertices. For each v € V| we define No(v) = {u € N(v) | deg(u) > 2} and Ny(v) = {u €
N(v) | deg(u) = 1}. We further partition H into two parts

Hy ={ve H[|Na(v)] < fo(k)} and Hy={veH]|[Na(v)] = fo(k)}.
Lemma 5.2.9. If Hy # () then there exists a locally minimal defensive alliance of size at

least k in G.

Proof. Let us assume that uy € Hy. We will show that there exists a locally minimal defen-

sive alliance of size at least k£ in G.

Case 1. Let us assume that |Ny(ug)| < |Na(ug)|. This implies that V(G) \ Nyi(ug) is a
defensive alliance. This is true because the number of neighbors of ug in V(G) \ Nq(uo) is

greater than or equal to the number of neighbors of uy in Ny(ug). So ug is protected; the
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other vertices are clearly protected. Now run Algorithm [2[on (G, V(G) \ Ni(ug), {uo}) and
suppose it returns D.

Subcase 1.1 If uy € Dy then at least degT("o) > k neighbors of uy are in Dy for its pro-
tection. That means, |Dy| > k.

Subcase 1.2 Suppose ug is not in Dy. Let D{ be the defensive alliance obtained at the
end of the while loop of lines 1-4 in Algorithm and all neighbors of uy in Dj, are overpro-
tected. This is why wg is deleted from Dj in line 5. We partition the vertices of defensive
alliance D{\{uo} into two parts: part P/ C N(ug) contains vertices adjacent to ug and part
P} contains vertices not adjacent to ug. Note that all the vertices in P/ have a degree of
at least two. Due to Lemma the vertices of P, will be there in a final locally minimal
defensive alliance as they are crucial in Dj,. Thus we have |P)°| < k — 1, otherwise we can
obtain a solution of size at least k by applying Algorithmon Dj. Furthermore, every vertex
in P}° has a degree at most 2k, or else we have a solution of size at least k. As every vertex
in P} has degree at most 2k, we prove that P contains at least one vertex u; with degree
at least fi(k) where fi(k) = k*'* for some large constant ¢;. Note that G[D}\ {uo}] contains
at most g — 1 connected components of size at least two, otherwise by applying Algorithm
on each of these defensive alliances, we will obtain a locally minimal defensive alliance of size
at least k. Moreover, every component of G[D{, \ {ug}] has diameter at most 4k* otherwise
by Theorem it is a yes instance. Therefore, if the maximum degree of G[Dj \ {uo}]
is f1(k) then we know that it contains at most O(k - f1(k)*”) vertices. We also know that
G[D) \ {uo}] contains at least O(fy(k)) vertices. Therefore, we get cfy(k) < 'k - fy(k)**

where ¢ and ¢ are two positive real numbers. This implies

c- fo(k)\ne

flk) = (S552)
Since we have fo(k) = k¥°", we can assume that fi(k) = k*'" for some large constant
¢1 < ¢g. As we have proved that the maximum degree of G[Dj \ {uo}] is fi(k), there must

exist a vertex in P with degree at least fi(k). Let us call that vertex u;.

We again run Algorithm[2Jon (G, D\ {uo}, {u1}). Let D] is a defensive alliance obtained
at the end of the while loop of lines 1-4 in Algorithm on (G, Dy \ {uo},{u1}). We can
apply the same argument as before and either obtain a locally minimal defensive alliance of

size at least k containing u; or a defensive alliance D]\ {u;} containing a vertex us of degree
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fo(k) = K*** for some large constant c,.

We repeat the same procedure k times. Let u, be the vertex obtained by the same pro-
cedure with a degree at least fi(k) = kE** for some large constant ¢;. Call Algorithm on
(G, D;_; \ {ug-1},ur) and suppose it returns Dy. If uy is in Dy then at least % >k
neighbors of u are in Dy, for its protection. That means we have a locally minimal defensive
alliance Dy of size at least k; so we are done. Suppose uy is not in Dy. Let Dj be the
defensive alliance obtained at the end of the while loop of Algorithm [2] We assume that
all neighbors of uy in D) are overprotected and this is why u;, is deleted. We partition the
vertices of defensive alliance Dj \ {uy} into two parts: P and Py*. A vertex x is in part
P/ if it is adjacent to all u;’s for i € [k] and a vertex z is in part Py* if it is not adjacent
to u; for any i € [k]. By Lemma|5.2.3] the vertices of Py* are crucial, and therefore these
crucial vertices will be there in the final locally minimal defensive alliance. If |Py*| > k, then
we have a locally minimal defensive alliance of size at least k, and we are done. Thus we
assume |Py*| < k. Consider a vertex x € P;"*. By the definition of P;"*, x is adjacent to k+1
vertices ug, uy, ..., u, which are outside the defensive alliance D), \ {ux}. As x is protected
in D; \ {ux} it must have at least k neighbors in D} \ {u;}. Thus deg,(z) > 2k + 1. Line 6
of Algorithmexecutes Algorithmon (G, D\ {ux}); suppose it outputs S. We prove that
at least one vertex of P/ will survive in S. For the sake of contradiction, assume that this
is false, that is, no vertex survive in S. Suppose vertex x in P/ is deleted last. It has at
most k neighbors, including itself, in the current defensive alliance as P,* contains at most
k — 1 vertices and k 4+ 1 neighbors outside the current defensive alliance. That means, x is
not protected, a contradiction to the fact that z is protected in D), \ {uy}. Therefore, the
locally minimal defensive alliance S obtained by Algorithm [2 contains a vertex of degree at

least 2k + 1; hence S is of size at least k.

Case 2. Let us assume that |Ny(ug)| > [Na(uo)
know that | N1 (ug)| > | Na(ug)|, without loss of generality, we can assume that deg(u;) = 1 for
i € [[554]]. Let us denote Ni(ug) = {uq, ua, . ... ’UU%J} C Ni(ug). Clearly, V(G)\ N (ug) is
a defensive alliance. Now run Algorithm |2jon (G, V(G) \ Ny (ug),{uo}). Suppose it returns
Dy.

. Suppose N(ug) = {u1,...,w}. As we

Subcase 2.1 If uy € Dy, then there exists a locally minimal defensive alliance of size
at least k.
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Subcase 2.2 Suppose uy € Dy. Let D{ be the defensive alliance obtained at the end of lines
1-4 in Algorithm [2]and all neighbors of ug in Dj) are overprotected. This is why we deleted
ug from D;, . As g is marginally protected in V/(G) \ Nf(ug) and Na(ug) € V(G) \ Ny (uo)
implies that Na(ug) C Dj. This is true because we cannot remove the neighbors of v, any-
more in V(G) \ Ny (ug) as g is marginally protected in V(G) \ Ny (ug). Next, we consider
the defensive alliance D = Dj \ (Ni(ug) U {uo}) where we get rid of degree one neighbors
of up from Dj. Just as in Subcase 1.2, we can partition the vertices of D{ into two parts
P ={w € N(up)ND{} and Py = {w & N(up) | w € Dj)}. Clearly, all the vertices in P}
are of degree at least 2, in fact, P"® = Na(ug). Also, we know that |Ny(ug)| = |Py°]| > fo(k).
From here on, we can apply the same argument as in Subcase 1.2 and obtain a locally min-

imal defensive alliance of size at least k. This proves Lemmam [1 Now let us focus on

vertices in the set H,. First, we make some simple observations about vertices in H;.

Observation 5.2.1. Suppose u € H; is contained in a locally minimal defensive alliance
S and u is overprotected. Then u can be made marginally protected in S by moving some

degree one neighbors of u from S to V'\ S.

Observation 5.2.2. A graph G has a defensive alliance D with a pair of adjacent marginally
protected vertices u,v € D if and only if G has a locally minimal defensive alliance S in

which u is marginally protected.

Lemma 5.2.10. Given a partition of V(G) into three parts A, B,C, we can check in poly-
nomial time if there is a defensive alliance D in G such that A C D and BN D = (). Also

if such a defensive alliance exists then it can be obtained in polynomial time.

Proof. We begin with the set D = AU C. Note that D may not be a defensive alliance.
We keep removing the unprotected vertices from D until all the vertices are protected. Let
D’ be the defensive alliance obtained by this method. If A C D’ then we return D’ as the
required defensive alliance. If A € D’ then one can see that there does not exist a defensive

alliance satisfying the given constraints. ]

Lemma 5.2.11. Given a vertex v € H, and Hy = 0, we can determine if there exists a

locally minimal defensive alliance containing v in FPT time.

Proof. We know that there exists a locally minimal defensive alliance containing v if and

only if there exists a defensive alliance S where v is a crucial vertex. Thus our goal here is
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to generate a defensive alliance where v is a crucial vertex. Due to Observation we
can assume that v is marginally protected in S. Also due to Lemma it is enough to
guess N(v) NS and N(N(v)NS)NS such that v will be marginally protected in S and also
have a marginally protected neighbor. Note that as v is marginally protected in S, guessing
S N Ny(v) already determines |[S N Ny(v)]. As Ni(v) is a set of degree one vertices, it also
determines SN N;(v) as any subset of Ny (v) of size | SN Ny (v)| will work. As |Ny(v)| < fo(k),
it implies that there are at most 2/0(%) guesses for SN Ny(v) and therefore for N (v)NS as well.
We partition the vertices in N(v) NS C Ny(v) into two types. Type 1 contains vertices of
degree at most 2fy(k). Type 2 contains vertices of degree more than 2f(k). Since we know
that Hy = (), all the vertices of type 2 are contained in H;. For every vertex w of type 1, we
can guess N(w) NS as the number of guesses required are 29°5(") where deg(w) < 2fo(k).
We also know that type 1 and type 2 vertices are bounded by fy(k). Since there are at most
fo(k) vertices of type 1, guessing N (typel) NS will require at most 2(2fo(k))* gyesses. Now
for every type 2 vertex w, since we know they are marginally protected in S, we need to
only guess No(w) N S. Since |Ny(w)| < fo(k), we need to make 2/0) guesses. Determining
No(w)NS will automatically determine | Ny (w)NS| and therefore Ny (w)NS as well. We have
made at most 2020(®)?*) guesses. Due to Lemma we can check in polynomial time
whether there exists such a defensive alliance S, which satisfies the guesses above. If the
above conditions are satisfied by S, that is, v € S, v is marginally protected, and v is crucial
then one can apply Algorithm [TJon S to get a locally minimal defensive alliance containing
v. Therefore we have an FPT algorithm to determine whether there exists a locally minimal
defensive alliance S containing v. Note that the running time of the algorithm is bounded
by 2(0(®)*R0M) - By substituting the value of fo(k), we get the running time of the algorithm
to be 2kk0(k) n®WM . This proves Lemma [5.2.11 O]

Given a graph G, we first check whether H, # (). If yes, then due to Lemma conclude
that we are dealing with a yes-instance. Therefore, we can assume that Hy = (). Next, for
each v € Hy, we check in FPT time whether there exists a locally minimal defensive alliance
containing v. If there exists a locally minimal defensive alliance containing v then conclude
that we are dealing with a yes-instance as deg(v) > 2k. Therefore, we can assume that there
does not exist any locally minimal defensive alliance containing a vertex from H;. So we
remove the vertices of H; from graph G. To do this, we create a weighted graph G’ from G in
the following way: G' = G — H; and w : V(G') — Z is a weight function where w(v) is equal
to the number of neighbors of v in H;. Note that G’ can be a disconnected graph. Suppose
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1, GY, ..., G are the connected components of G'. We observe that D C V() is a defensive
alliance in G if for all v € D, we have degp(v)+1 > degy (g, p(v) +w(v). Given a connected
component G, if there exists a vertex v € V(G;) such that dege (v) +1 < w(v) then we
update G’ to G, —{v} and update the weight of the vertices in G, —{v} to w(u) = w(u)+1 for
all u € N(v), otherwise we keep the same weight. This is true because such a vertex v cannot
be part of any locally minimal defensive alliance in G. Now let us assume that after this

“reduction" procedure, we get G, G5, ..., G}, I' <1, as nonempty connected components.

Claim 5.2.2. For each i € [I'], V(GY) forms a defensive alliance in G.

Proof. Let v € V(GY). We know that degy gr)(v) +1 > w(v), otherwise v must have been
deleted from G7. Note that w(v) = degy (g)\vgr)(v) by construction. Therefore, we have
degy @y (v) + 1 > degy e (v) for all v € V(GY). This completes the proof of the

claim. =

Note that we have deleted the vertices v for which we know that there is no locally minimal
defensive alliance containing v. Now, one can check if diam(G[V (GY)]) > 4k? for some 4. If
yes then conclude that we are dealing with a yes-instance due to T heorem Therefore
we can assume that diam(G[V(GY)]) < 4k* for all i. We also know that A(GY) < 2fo(k).
This means that we have a weighted graph where a function of k£ bounds the size of each
connected component. From here, one can do brute force to find the largest locally minimal
defensive alliance in each GY. Finally, we can add the sizes of the largest locally minimal
defensive alliances from different connected components, and if the sum is at least k& then

conclude that we are dealing with a yes-instance.

5.2.2.2 A(G) is small:

Given a graph G, if we know that A(G) is at most k*°" for some constant ¢, and also
that the diameter of each connected component is at most 4k? then clearly each connected
component size is bounded by a function of k£ only. As explained in previous paragraph, we

can easily get a locally minimal defensive alliance of size at least k in FPT time.

109



5.2.3 Proof of Theorem [5.1.1]

Given a graph G, we first check if the diameter of G is at least 4k%. This can be done in
polynomial time. If yes, then due to Theorem [5.2.4]it is a yes instance. Hence, we assume
that the diameter of G is less than 4k?. We find a vertex v in G with maximum degree A(G).
As explained in Section [5.2.2.1] if A(G) > k*°" for some constant ¢y then we can solve the
problem in FPT time. Finally, if A(G) < kF" then due to Section |5.2.2.2] we know how
to find a locally minimal defensive alliance of size at least k in FPT time. Let us calculate
the total time required to solve the problems in Section and Section One

can observe that the worst case time complexity is obtained when we solve the problem by

brute force technique. Therefore the worst case time complexity is essentially the number of

subsets of connected components whose maximum degree is bounded by 2 fy(k) and diameter

(k)4k2

is bounded by 4k2. That is, we need to consider at most 2/0 many subsets. Therefore,

O(k3)
we get the worst case time complexity to be ok no

). This proves Theorem|5.1.1

5.3 Kernels for LOCALLY MINIMAL DEFENSIVE ALLIANCE
restricted to Cs-free and C)-free graphs

In this section we give improved kernels for LOCALLY MINIMAL DEFENSIVE ALLIANCE
restricted to Cs-free and C)-free graphs, when the parameter is the solution size k. The

following lemma is the basis for our kernelization algorithm.

Lemma 5.3.1. Let G be a Cs-free graph with a minimum degree at least 2. If A(G) > 4k?

then G has a locally minimal defensive alliance of size at least k.

Proof. Suppose d(u) = 4k?. Run BFS(G,u) to obtain the BFS tree T rooted at u and to
get level for each vertex in G. Let L; be the set of vertices at level ¢ of T. Run Algorithm
on (G,V(G),u) and suppose it outputs D. If u € D then clearly D is a locally minimal
defensive alliance of size at least 2k? as the degree of u is 4k* and w is protected in D.
Consider the case where u is not in D. Let D’ be the defensive alliance obtained at line 5
of Algorithm As wu is protected in D’ at least 2k? of its neighbours are in L; N D’. All
neighbors of u in L; N D’ are overprotected, this is why u is deleted from D’ in line 6. As
G is triangle-free and 6(G) > 2, every x € L; N D’ has least one neighbour in Ly N D'. We
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know every vertex from level 2 or higher that survives in the defensive alliance obtained by
Algorithm [2|is crucial and they remain in the final locally minimal defensive alliance. This
implies that there are at most k — 1 vertices in D' N Ly, otherwise we have a solution of size
at least k. Note that there are at least 2k? vertices in D' N Ly, at most k — 1 vertices in
Ly N D" and each vertex in D’ N Ly has at least one neighbor in Ly, N D’. By the Pigeonhole
principle there exists a vertex w € Ly N D’ of degree at least 2k. As w is crucial it is in the
final solution. Moreover, as d(w) > 2k, the final locally minimal defensive alliance is of size
at least k.

Lemma 5.3.2. Let G be a Cy-free graph with a minimum degree of at least 2. If A(G) >

4k? — 2k then G has a locally minimal defensive alliance of size at least k.

Proof. Let d(u) > 4k* — 2k. Run BFS(G,u) to obtain the BFS tree T rooted at u and to
get level for each vertex in G. Run Algorithm on (G,V(G),u) and suppose it outputs D.
If w € D then clearly D is a locally minimal defensive alliance of size at least 2k* — k as the
degree of u is 4k? — 2k and w is protected in D. Consider the case where u is not in D. Let D’
be the defensive alliance obtained at line 5 of Algorithm As u is protected in D’ at least
2k? — k of its neighbours are in L; N D’. All neighbors of v in L; N D’ are overprotected, this
is why u is deleted from D’ in line 6. Now let us focus on G[L; N D’]. It may be noted that
the maximum degree of a vertex in G[L; N D'] is at most 1 as G is Cy-free. As 6(G) > 2, all
isolated vertices in G[L;ND’] must have at least one neighbour in LoND’. Every vertex from
level 2 or higher that survives in the defensive alliance obtained by Algorithm [2|is crucial
and it remains in the final locally minimal defensive alliance. Thus we assume there are at
most k£ — 1 vertices in D’ N Lo, otherwise we have a solution of size at least k. Furthermore,
every vertex in D’ N Ly has a degree at most 2k, or else we have a solution of size at least k.
This implies that there are at most 2k? — 2k vertices in G[N(u) N D’] which have neighbour
in L, N D’. Note that there are still k& vertices that have no neighbor in L, N D’; so they
must have at least one neighbor in Ly N D’. As G[N(u) N D'] has maximum degree one, we
must have at least £ isolated edges in G[D'\ {u}]. As these £ isolated edges in G[D"\ {u}]
are connected defensive alliances such that the closed neighborhood of one defensive alliance

does not intersect the other, we get a locally minimal defensive alliance of size at least k. [
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5.3.1 Proof of Theorem

Proof. It A(G) > 4k? or diam(G) > O(k) then we get a yes instance due to Lemma
Lemmal5.3.2|and the Lemmal5.4.4|. Therefore, we assume G has A(G) < 4k? and diam(G) <
O(k) which produces a kernel of size k9*). O

5.4 FPT algorithm for LOCALLY MINIMAL DEFENSIVE AL-
LIANCE on planar graphs

It is proved in [71] that the LOCALLY MINIMAL DEFENSIVE ALLIANCE is NP-complete
in planar graphs via a reduction from MINIMUM MAXIMAL MATCHING in a cubic planar
graph. In this section, we design an FPT algorithm for LOCALLY MINIMAL DEFENSIVE
ALLIANCE on planar graphs with 6(G) > 2. We use a win/win approach to design an FPT
algorithm for LOCALLY MINIMAL DEFENSIVE ALLIANCE on planar graphs. For n € N, by
[n] we denote the set {1,2,...,n}. For a positive integer ¢, a t x t grid H; is a graph with
vertex set {(z,y) : x,y € [t]} and two different vertices (z,y) and (2/,y’) are adjacent if
and only if |z — 2| + |y — ¢/| = 1. The triangulated grid I'; is obtained from the grid B; by
adding the edges (z 4+ 1,y), (z,y+ 1) for all 1 < z,y <t —1 and additionally making vertex
(t,t) adjacent to the whole border of H;. Theorem gives the relationship between the

treewidth and the size of a triangulated grid as a contraction.

Theorem 5.4.1. [5/] (Planar excluded grid theorem for edge contractions). For
every connected planar graph G and integer t > 0, if tw(G) > 9t + 5 then G contains I'y as
a contraction. Furthermore, for every ¢ > 0 there exists an O(n?) algorithm that, given a
connected planar n-vertex graph G and an integer t, either outputs a tree decomposition of

G of width (94 €)t +5 or a set of edges whose contraction in G results in I'y.

We now obtain the following result.

Lemma 5.4.2. Let G be a planar graph of minimum degree at least 2 which contains a
triangulated grid T',) 7, , as a minor obtained by only edge contraction operations. Then

there always exists a locally minimal defensive alliance of size at least k.

Proof. The proof of Lemma is similar to that of Lemma Given that G can be
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transformed to I'; after a sequence of edge contractions. Suppose that the vertices of I'; are
labelled (4, j) where 4,5 € [t]. Consider the set C' = {(4z + 2,4y +2) | 0 < z,y < £} C
V(T'y). See Figure for an illustration; the vertices of C" are shown in red. Every (red)
vertex in I'; is either an original vertex of G or obtained by contracting some edges of G.
We obtain a set C' C V(@) from C’ as follows. For each (red) vertex (i,7) in C’, if the red
vertex is an original vertex, then include it in C’ if the red vertex is obtained by contracting
some edges of GG, then arbitrarily include in C' an endpoint of one of the contracted edges.

Run Algorithm on (G,V(G),C). Let D¢ be a defensive alliance obtained at the end of

Figure 5.4: Example of a triangulated grid I'y4. Note that a blue edge denotes that the
vertex is adjacent to all the vertices inside the blue boundary. The set of vertices colored
red forms C’.

the while loop of lines 1-4 in Algorithm Note that Do may not be a connected defensive
P

alliance. Let Sy, 5%, ...,S, be connected components of D¢ such that |J S; = D¢. This
i=1

implies that D¢ will generate a partition of C into C4, Cy, ..., C, such that C; C S;.

Claim 5.4.1. Line 6 of Algorithm@ rUnS Algom'thm on (G,S;) and outputs a locally
minimal defensive alliance S| such that |S}| > max {2, |C;| — 1} for all 1 <i <p.

Proof of Claim: As we have §(G) > 2, we have |S!| > 2. Therefore let us focus on C;’s such
that |C;| > 4. Let C; ={c1,¢a,...,¢4}. Then dg(cs,¢;) >4 forall 1 <s,t<g—1ands#t
due to choice of C. Note that as G[S;] is connected, it implies that G[S;] contains at least
q — 1 crucial vertices in the defensive alliance S;. This is because any path between ¢, and
¢ in G[S;] contains a vertex u such that d(u,C;) > 2 for all 1 <7 < ¢ — 1. Due to Lemma
we have |S]| > ¢ — 1. This completes the proof of the lemma.
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If we set t = 10v/k + 4 then |C| > 2k. Tt implies that U S! is a locally minimal defensive

alliance of size at least k. This completes the proof of the lemma. O

To design a dynamic programming algorithm on a given tree decomposition of the input

graph, we use the following theorem.

Theorem 5.4.3. [71] Given an n-vertex graph G and its nice tree decomposition T of width

at most w, the size of a maximum locally minimal defensive alliance of G can be computed
in time O* (18 APW)).

In the next lemma, we improve the bound given in Theorem |5.2.4] for graphs with

minimum degree at least two. Note that the proof is similar to Lemma|5.2.8

Lemma 5.4.4. Let G be a graph such that §(G) > 2 and S be a connected defensive alliance
of diameter at least 8k" where k' is any positive integer then there exists a locally minimal

defensive alliance of size at least k.

Proof. As diam(G[S]) > 8k', there exists a pair of vertices wy and wgy such that d(wg, wsg) =
8k" in G[S]. Let P =< wg,wy,...,wspr > be a shortest wo-wgy path in G[S]. Set C' =
{wy | 0 < i <2k}, Run Algorithm on (G,S,C). Let S(C) be the defensive alliance
obtained at the end of the while loop of lines 1-4 in Algorithm [2| Note that S(C) may
not be a connected defensive alliance. Suppose S(C4), S(Cy),...,S(C,) are the connected
components of S(C') which generates a partition of C' into p parts Cy,Cs,. .., C, such that
C; C S(C;) for all j € [p]. Line 5 of Algorithmlexecutes Algorithmon (G,S(C)), that
is, it executes Algorlthmlon (G,S(Cy)) for all j € [p].

Claim 5.4.2. Line 5 of Algorithm@ TUNS Algom'thm on (G, S(C;)) and outputs a connected
locally minimal defensive alliance S; such that |S;| > max {2, |C;| — 1} for all j € [p].

Proof. As we have §(G) > 2, any (locally minimal) defensive alliance in G must be of size
at least 2. Therefore, we get |S;| > 2. We now prove that Algorithm [1] Ion input instance
(G, S(C;)) returns a connected locally minimal defensive alliance \S; of size at least |C;| — 1.
Suppose C; = {c1,...,¢.}, ¢ > 2. We know d(¢, ¢i1) > 4 and d(wy, ¢;) < d(wo, ci41) in
G[S(Cj)] forall 1 <1 < g—1. As G[S(C})] is connected, a shortest path between ¢; and ¢;44
in G[S(C;)] contain a vertex, say x, such that z € S(C;) \ N[C;]. By Lemma ris a

crucial vertex in S(C;). As Cj; has g vertices, we have at least ¢ — 1 crucial vertices in S (C).
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By Lemma 2| the locally minimal defensive alliance .S; returned by line 4 of Algorithm [2) I
contains all ¢ — 1 crucial vertices of S;. Thus we have |S;| > ¢ — 1. This completes the proof

of the claim.

P
As the closed neighbourhood of one S; does not intersect others and |C| > 2K/, we get |J 5;
j=1
is a locally minimal defensive alliance of size at least k’. This completes the proof of the

lemma. O

Corollary 5.4.5. Let [ and k be positive integers and Sy, Sy, ..., be a set of connected
defensive alliance in G with §(G) > 2 such that N[S;]NS; = @ for all i # j and ¢, 7 € [l]. If

8.(d; + 1) > diam(G]S;]) > 8.d; for some integer d; such that Z d; > k then G has a locally

minimal defensive alliance of size at least k. Also, such a locally minimal defensive alliance

can be obtained in polynomial time.

Proof. Due to Lemma [5.4.4] given a defensive alliance S; one can obtain a locally minimal
defensive alliance of size at least d; for d; > 1. Note that when d; = 0, one can simply apply
algorithm [1fon S; to get a locally minimal defensive alliance of size at least 2. It is easy

to see that the union of such locally minimal defensive alliances is also a locally minimal

defensive alliance as N[S;] N S; = 0 for all i # j and 7,5 € [[]. Since > d; > k, we get a
i=1
locally minimal defensive alliance of size at least k. [

Finally, to design an FPT algorithm when parameterized by the solution size k, we prove

the following lemma.

Lemma 5.4.6. Let G be a planar graph with a minimum degree of at least 2. If A(G) > ck®
where ¢ is a sufficiently large constant then there exists a locally minimal defensive alliance

of size at least k.

Proof. Let v be any vertex such that d(v) > ck®. Run BFS(G,v) to obtain the BFS tree T
rooted at v and to get the level for each vertex in G. We will first run the Algorithm [2]on
(G,V(G),v) and suppose it outputs Dy. If v € Dy then at least d(”

in Dy for its protection. Therefore Dy is a locally minimal defenswe alliance of size at least

> k neighbours of v are

k. Consider the case v is not in Dy. Let D; be the defensive alliance obtained at the end of
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Figure 5.5: A planar drawing of the vertices in (DN L;)U{v}. The vertices colored green are
inside the defensive alliance, and the vertices colored red are outside the defensive alliance.

the while loop of lines 1-4 in Algorithm [2| and all neighbors of v in D; are overprotected.
This is why v is deleted from Dy. As we have discussed before, the vertices in D; N L>, are
crucial. Therefore if Dy N L>, contains more than k£ — 1 vertices or any of them has a degree
more than 2k, then we have a locally minimal defensive alliance of size at least k. So we
assume |Dy N Lso| < k — 1 and every vertex in Dy N Lxy has degree less than 2k. Now we
consider the defensive alliance D = D, \ {v}. As v was protected in Dy, D N L, contains at

least L%j vertices.

Case 1. Let us assume that there exists a vertex w in D N Ly such that it has at least
34k neighbours in D N L;. In this case, we run Algorithm on (G, D,{w}) and suppose it
outputs Dy. If w € Dy then at least @ > k neighbours of w are in D, for its protection.
Therefore Dy is a locally minimal defensive alliance of size at least k. Consider the case
w is not in Dy. Let D3 be the defensive alliance obtained at the end of the while loop
of lines 1-4 in Algorithm |2| and all neighbors of w are overprotected in Djz. This is why
w is deleted from Dj. Let us denote Dy = D3 \ {w}. Since w was protected in D3 and
it had at most £ — 1 neighbours in D N L>,, Dy must contain at least 16k vertices from

set N(w) N N(v). As D, may not be a connected defensive alliance, let us assume that
!

Dy = | Dy; where Dy, Dys, ..., Dy be a set of connected defensive alliance in G such that
i=1
N[Dy) N Dy; =0 for all ¢ # j and ¢, j € [I]. Due to planarity of the graph, one can observe

that diam(G[Dy]) > max{1,|Dy N N(w) N N(v)| — 1}. See Figure for an illustration.

!

This shows that ) diam(G[Dy]) > 8k. Due to Corollary |5.4.5| a locally minimal defensive
i=1

alliance of size at least k exists.
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Case 2. We assume that every vertex in DM L; has at most 34k neighbors in the set DN L;.
As |D N Lsy| < k — 1, the maximum degree of graph G[D] is at most 35k. As D contains
at least % vertices and A(G[D]) < 35k, one can greedily construct a 4-scattered set C' in a
graph G[D] of size at least c'k? for a large constant ¢/, assuming that c is sufficiently large.
Next run Algorithmon (G,V(G),C). Let D¢ be a defensive alliance obtained at the end
of the while loop of lines 1-4 in Algorithm [2| Note thag D¢ be a defensive alliance. Let

S1,S2,...,S, be connected component of D¢ such that |J S; = D¢. This implies that D¢
i=1
will generate a partition of C' into Cy,Cy, ..., C, such that C; C ;.

Claim 5.4.3. Line 6 of Algorithm@ rUuns Algom'thm on (G,S;) and outputs a locally

minimal defensive alliance S} such that |Sj| > max {2, %} forall1 <i<np.

Proof. Clearly, as minimum degree of G is at least 2 implies that |S/| > 2. Therefore let
us focus on Cj’s such that |C;| > 70k. Let C; = {c1,¢2,...,¢,}. We have d(cy,cj) > 4 for
all 1 > ¢, 7' > g and 7 # j' in G[S;] as C; is a 4-scattered set in G[S;]. All the vertices in
S; \ N[C;] are crucial in S; due to Lemma To get a lower bound on the number of
crucial vertices, we make the following observation. Let us consider the subgraphs G[N[C}]]
as an induced subgraph of graph G[S;]. Clearly the induced subgraph G[N|[C;]] have exactly
¢ many connected components. For any vertex subset {zy,zs,...,2;} € S; \ N[C;], the
induced subgraph G[N|[C;] U {x1,x2,...,2;}] will contain at least max{1,q — (35k)(} con-
nected components as A(G[S;]) < 35k. As we know that G[S;] is connected, we must have
|S; \ N[Ci]| > =L which is essentially the number of crucial vertices in the defensive alliance

35k

S;. Therefore we get that |S!| > % This proves the claim.

P b,
Due to the claim above, we see that 2:1 |SI| > 231 % > k as ¢ is a large constant. Also, it
= i=

p
is easy to see that |J S is a locally minimal defensive alliance. ]
i=1
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5.5 LOCALLY MINIMAL DEFENSIVE ALLIANCE Parameter-
ized by Treewidth

This section presents an XP-algorithm for LOCALLY MINIMAL DEFENSIVE ALLIANCE prob-

lem parameterized by treewidth. We prove the following theorem:

Theorem 5.5.1. Given an n-vertex graph G and its nice tree decomposition T' of width at

most k, the size of a mazimum locally minimal defensive alliance of G can be computed in

O (18 n**+19) time.

Let (T, {X:}tev(r)) be a nice tree decomposition rooted at node r of the input graph G.
For a node t of T, let V; be the union of all bags present in the subtree of T rooted at t,
including X;. We denote by G, the subgraph of GG induced by V;. Here we distinguish not
only if a vertex is in the solution or not, but if it is in the solution we also distinguish if it is
marginally protected or not. A coloring of bag X, is a mapping f : X; — {b, w,r} assigning

three different colours to vertices of the bag. We give intuition behind the three colours.

e White, represented by w. The meaning is that all white vertices have to be contained

in the partial solution in Gj.

e Black, represented by b. The meaning is that all black vertices have to be contained in
the partial solution in Gy; additionally, all black vertices must be marginally protected

in the final solution.

e Red, represented by r. The meaning is that all red vertices are not contained in the

partial solution in Gj.

For a node t, there are 3% colourings X;. Now, for each node ¢ in T, we construct a table
dp(f,p,a,v,a,m, (3, 8%,v) € {true, false} where f is a colouring of X, p is a vector of length
n such that
‘ Oorl if v; € Xy and f(v;) € {b,w}

p(t) = * otherwise;
a and v are vectors of length n, and their ith coordinates are positive only if v; is in
X; and it is coloured b or w; «,m, 3,8* and ~ are integers between 0 to n. We set

dp(f,p,a,v,a,m, 3, 5% ) = true if and only if there exists a set A; C V; such that
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ca=4={veVi: f(v) € {bw}}
. fTHb,w} = AN X, = A, which is the set of vertices of X; colored black or white.

. the 7th coordinate of vector p is

1 if v; € Xy, f(v;) € {b,w} and v; has a black neighbour in A,
p(i) =40 if v; € Xy, f(v;) € {b,w} and v; has no black neighbours in A,

* otherwise

. the ith coordinate of vector a is

: da,(vi)  ifv; € Xy and f(v;) € {b,w}
a(i) =

0 otherwise

That is, a(i) denotes the number of neighbours of vertex v; in A; if v; € X; and

fv) € {b,w}.
. the ith coordinate of vector v is

(i) = dy,(vi)  ifv; € Xy and f(v;) € {b,w}

0 otherwise.

That is, v(i) denotes the number of neighbours of vertex v; in V; if v; € X; and

flv) € {b,w}.

. 7 is the number of vertices v € A; that are protected, that is, d4,(v) > dc(g)fl.

. [ is the number of black vertices in A;.

. B* is the number of black vertices v in A; such that N(v) C V; and dy, (v) = (%1
Thus §* is the number of black vertices v in A; that are marginally protected when
all its neighbours are introduced in G;. The intuition here is that we want every back

vertex to be marginally protected when all its neighbours are introduced.

. 7y is the number of vertices in A; who has a black neighbour. In other words, ~y is the

number of good vertices in A;.
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We compute all entries dp;(f, p,a, v, a,m, 3, 8%, ) in a bottom-up manner. Since tw(7T) < k,
there are O(3% - 2% . n¥ . nk . (n +1)%) = O(6*n?*5) possible tuples (f,p,a,v,a,, 3, 5%, 7).
Thus, to prove Theorem it suffices to show that each entry dp,(f,p,a,v,«a,m, 3, 5*,7)
can be computed in O(3%n**5) time, assuming that the entries for the children of ¢ are

already computed.

Lemma 5.5.2. For a leaf node t, dp,(f,p,a,v,a, 7,3, 5*,7) can be computed in O(1) time.

Proof. For leaf node t we have that X; = (). Thus dp.(f,p,a,v,a,x, 3, % ~) is true if and
onlyif f=0,p=0,a=0,v=0,a=0,7r=0,3=0, *=0and v =0. These conditions
can be checked in O(1) time. O

Lemma 5.5.3. For an introduce node t, dp,(f,p,a, v, o, B, 5*,v) can be computed in O(1)

time.

Proof. Suppose t is an introduce node with child ¢ such that X; = Xy U {v;} for some
v; ¢ Xy. Let f be any coloring of X;. We consider three cases:

Case (i): Let f(v;)) = r. In this case dp(f,p,a,v,a,m (3, 5% ) is true if and only if
dpt'(f|Xt/7p7 a, V,7 «, T, 57ﬁ*77) is true where

v'(j)+1 if j #1i,v; € Xy, f(v;) € {b,w} and v; € Nx,(v;)

v/'(7) otherwise

Case (i1): Let f(v;) = b. Here dp,(f,p,a,v,a,m, 3, 5* 7) is true if and only if there exist a
tuple (f’,p’,a’,v’,a’,ﬁ’,ﬁ’,ﬁ*',fy’) such that dpt/(f’,p’,a’,v’,a’,ﬂ’,ﬁ’,ﬁ*',fy’):true, where

L flxagey = flx,;

) 1 if v; € Xy, f(vj) € {b,w} and v; € N(v;)
Pl =
p'(j) otherwise
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a(j)+1 if j #1i,v; € Xy, f(v;) € {b,w} and v; € Nx,(v;)
a(j) = |[Na(vy)| if j =1
a'(j) otherwise

where A = A; N X,.

4.
v'(j) +1 it j #1i,v; € Xy, f(v;) € {b,w} and v; € Nx,(v;)
v(7) = | INx, (vi)] it j =1
v'(j) otherwise
5. a=d +1;

6. m = ' + [; here [ is the cardinality of the set

{v € X1 £v) € fbwh () <

That is, to compute 7 from 7’ we need to add the number [ of vertices v; € X; which

are not protected in X, but protected in Xj.

7. 6=0+1,
8. B*=p"+6
where 0 is the number of black vertices v; € X; such that a’(j) # (%W or v'(j) #

d(v;) but it satisfies the conditions a(j) = [%] and v(j) = d(v;).

9. v=7"+Nvj € Al p(j) =1 but p'(j) = 0},

Case (iii): Let f(v;) = w. Here dp(f,p,a,v,a,m, 3, *,7) is true if and only if there exist a
tuple (f/’ p/7 a/v VI7 O.//, 7T/a 6/7 ﬂ*la ’y/) such that dpt’(f/’ p/7 a/a VI7 (X/, 7T/a ﬁlv 6*/a ,y/):truea where

L flxaqoy = f';

2.
p'(j) ifj#i
p(j) =141 if j =7 and v; has a black neighbour in X,
0 if 7 =4 and v; has no black neighbours in X}
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a(j)+1 if j #1i,v; € Xy, f(v;) € {b,w} and v; € Nx,(v;)
a(j) = |[Na(vy)| if j =i
a'(j) otherwise

where A = A; N X,.

4.
v'(j)+1 if j #14,v; € Xy, f(vj) € {b,w} and v; € Nx,(v;)
v(7) = § INx, (vi)] it j =1
v'(j) otherwise
5. a=d +1;

6. m = 7’ + [; here [ is the cardinality of the set

{Uj € X, | flvy) € {byw},a'(j) < M NS dg(vj) — 1}'

That is, to compute 7 from 7’ we need to add the number [ of vertices v; € X; which

are not protected in Xy but protected in X;.
7. 0=0"

8. B*=p"46
where ¢ is the number of black vertices v; € X; such that a’(j) # [%] or v'(j) #

d(v;) but it satisfies the conditions a(j) = f%] and v(j) = d(v;).

9. v =+"+1if v; is adjacent to a vertex in X; which is coloured black; otherwise v = /.

For introduce node ¢, dp,(f,p,a,v,a,w, 3, *,7) can be computed in O(1) time as there is
only one candidate of such tuple (f/,p’,a’,v', o/, 7', 3, 5%, ). ]

Lemma 5.5.4. For a forget node t, dp,(f,p,a,v,«a,m, B, 5% ) can be computed in O(n)

time.

Proof. Suppose t is a forget node with child ¢ such that X, = Xy \ {v;} for some v; € X, .
Here dp,(f, p,a, v, a, m, 3, 3%, 7) is true if and only if dp, (', p,a’, v/, o/, 7', 3, B*,+) is true,
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where

—_

. f/ - fvi—>b; fvi—m) or fvi—w-

p'(j) ifj#i

p(j) =
* if j =1

3. a(j) =a'(j) for all j # ¢ and a(i) = 0;

4. v(j) =v/'(j) for all j # i and v(i) = 0;

5. a=do;
6. m=7';
7. 8=70"
8. % =p";
9. v=+

There are n + 1 choices for a’(i) and v/(i) each. Thus the lemma follows as there are O(n)
candidates of such tuples (f/,p’,a’,v', o/, 7', 8", 3*,7'). This completes the proof of the

lemma. O

Lemma 5.5.5. For a join node t, dp;(f,p,a,v,a,m, 3, 8%,7) can be computed in O(3*n3++5)

time.

Proof. Suppose t is a join node with children ¢; and ¢5 such that X; = X;, = X;,. Then
dpt(fv p,a,v,q,T, Ba 5*7 7) is true if and Only if there exist (f17 P1,a1,Vy, 0, T, ﬁla /BT7 ’Yl) and

(f2, P2, @2, Vo, 2, o, fa, 5, 72) such that dpy, (f1, p1, a1, vi, a1, 71, b1, 67, 71) and dpy, (f2, P2, ag, Vo, o, T2, b5
are true, where

L f=fi= [
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2. p(i) =1if p1(i) = 1 or p2(i) = 1;

3. a(i) = a;(i) + ax(i) — da(v;) for all v; € A, and a(i) = 0 if v; ¢ A where A = {v €
X | f(v) € {b,w}};

4. v(i) = vi(i) + vo(i) — dx,(v;) for all v; € A, and v(i) = 0 if v; ¢ A;
5. a=a;+ay —|Af;

6. 7T:7T1—|—7T2—l1+l2;

where [; is the cardinality of the set

. d (4 -1 . d Vi -1
[oyeala) = WL () » )21
and [y is the cardinality of the set
. d V; -1 . d (o -1 . d V; -1
[y eala) < WL g < WL ) 5l 21y

To compute 7 from 7, + 5, we need to subtract the number of those v; which are pro-
tected in both the branches and add the number of vertices v; which are not protected

in either of the branches t; and ¢, but protected in ¢.
7. 8=p+B—{ve Al flv) =0}

8. " = [B7 4 B5 + 01 + 02 — 012
Here 6, is the number of black vertices v; in X such that a;(j) # [dc—w or vi(j) #
d(v;) but it satisfies the conditions a(j) = (dcv—])l] and v(j) = d(v;). Similarly, J, is
the number of black vertices v; in X; such that either as(j) # fM1 or va(j) # d(v;)
but it satisfies the conditions a(j) = (%} and v(j) = d(vj). Finally ;2 is the
number of black vertices v; in X; such that a;(j) # (M} or vi(j) # d(vj) and

2

ax(j) # [%1 or vo(j) # d(v;) but it satisfies the conditions a(j) = [dG o) 1 and

v(j) = d(v;).
9. vy=m+7—{ved|pi(v)=pav) =1}
For join node ¢, there are at most 3* possible pairs for (p1, p2) as (p1 (i), pz( )) € {(1,0),(0,1),(1,1)}

when p(i) = 1 and (py1(4), p2(i)) = (0,0) when p(i) = 0; there are n* possible pairs for

(a1,a2) as ay is uniquely determined by ay; there are n* possible pairs for (v, vy) as vy is
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uniquely determined by vy; n+ 1 possible pairs for (v, as); n+ 1 possible pairs for (7, m5);
n + 1 possible pairs for (81, 82); n + 1 possible pairs for (55, 53); and n + 1 possible pairs
for (71,72). In total, there are O(3¥n?*5) candidates, and each of them can be checked in
O(1) time. Thus, for join node ¢, dp;(f, p,a, v, o, 7, 3, 3*,7) can be computed in O(3¥n+5)
time. O

At the root node r, we look at all records such that dp, (0,0, 0,0, a, 7, 3, 5*,v)= true, § = B*
(that is, all black vertices in the solution are marginally protected) and @ = 7 = ~ (that
is, every vertex in the solution is protected and has a black or marginally protected neigh-

bour). The size of a maximum locally minimal defensive alliance is the maximum « satisfying

dpr(q)a Q)a 97@70[777-7676*77): true7 a=7T=7° and B - B*

Remark. The above algorithm implies that LOCALLY MINIMAL DEFENSIVE ALLIANCE

can be solved in polynomial time on trees.

5.6 Closing Remarks and Future Directions

We showed that LOCALLY MINIMAL DEFENSIVE ALLIANCE is FPT when parameterized
by solution size. On the Cs-free and Cj-free graphs with minimum degree at least 2, we
have provided a kernel of size k°*). When restricted to planar graphs with minimum degree

O It remains open

at least 2, we have designed an algorithm with running time kORI,
whether LOCALLY MINIMAL DEFENSIVE ALLIANCE admits a kernel of polynomial size. Also
it would be interesting to know if our ideas can be used to decide if GLOBALLY MINIMAL

DEFENSIVE ALLIANCE is FPT when parameterized by solution size.
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Chapter 6

Globally Minimal Defensive Alliance

A defensive alliance in an undirected graph G = (V, E) is a non-empty set S of vertices
satisfying the condition that every vertex v € S has at least as many neighbours (including
itself) in S as it has in V' \ S. We consider the notion of global minimality in this chapter.
A defensive alliance S is called a globally minimal defensive alliance if no proper subset
is a defensive alliance. Given an undirected graph G and a positive integer k, we study
GLOBALLY MINIMAL DEFENSIVE ALLIANCE, where the goal is to check whether G has
a globally minimal defensive alliance of size at least k. This problem is NP-hard but its
parameterized complexity remains open until now. The goal of this chapter is to provide new
insights into the complexity of GLOBALLY MINIMAL DEFENSIVE ALLIANCE parameterized
by the structure of the input graph. We show that the problem is FPT parameterized by the
neighbourhood diversity of the input graph. The result for neighborhood diversity implies
that the problem is FPT parameterized by vertex cover number also. We prove that the
problem parameterized by the vertex cover number of the input graph does not admit a
polynomial compression unless coNP C NP /poly. We show that the problem is W[1]|-hard
parameterized by a wide range of fairly restrictive structural parameters such as the feedback
vertex set number, pathwidth, treewidth and treedepth. We also prove that, given a vertex
r € V(G), deciding if G has a globally minimal defensive alliance of any size containing

vertex 1 is NP-complete. This chapter is based on the paper [63].
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6.1 FPT algorithm parameterized by neighbourhood di-

versity

In this section, we show that when parameterized by neighbouhood diversity, the problem
of finding a largest globally minimal defensive alliance is fixed-parameter tractable. In this

section, we prove the following theorem:

Theorem 6.1.1. GLOBALLY MINIMAL DEFENSIVE ALLIANCE can be solved in time kO*°).
n®W where k is the neighbourhood diversity of the input graph.

We reduce the problem of finding a largest globally minimal defensive alliance to an
integer linear programming optimization with k variables. Since integer linear programming
is fixed parameter tractable when parameterized by the number of variables [99], we conclude

that our problem is FPT when parameterized by the neighbourhood diversity k.

6.1.1 ILP formulation

Let G be a connected graph such that nd(G) = k. In this section we assume that we have
the partition of V(G) into sets of type classes C1, ..., Cy. We assume k > 2 since otherwise

the problem becomes trivial. We prove the following lemma.

Lemma 6.1.2. Suppose Sy, Sy C V(G) are such that |S; N Cy| = |Sa NG| for all i € [k]. If
S1 is a GMDA in G then Ss is also a GMDA in G.

Proof. Suppose S is a globally minimal defensive alliance in G. For each i € [k], the vertices
in S; N C; and Sy N C; have the same neighbourhood in G as the vertices in C; have the
same neighbourhood in G. Therefore S is also a globally minimal defensive alliance in G.
Similarly, given S5 is a globally minimal defensive alliance in G, it is easy to prove that S;

is also a globally minimal defensive alliance in G. O

Let x; = |C; N S| where S is a globally minimal defensive alliance. We define the following

sets:

COI{Cz’$Z:O}7 ]0:{Z|Cz€CO}
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Clz{CZ|ZEZ:1}, Ilz{Z|CZEC1}
Coo={Ci | 2; >2}; Iy ={i| Ci € Cso}

Our goal here is to find a largest globally minimal defensive alliance S of G, with C;N.S = ()
when C; € Co, |C; N S| =1 when C; € C; and |C; N S| > 2 when C; € C>2 where Cy, C; and
C>o are given. By Lemma|6.1.2 the variables x; determine S uniquely. The objective is to

maximize the sum
L]+ ) xi[C; € Cx
1€[k]
under the condition 2 < z; < |C;| = n; for all ¢ € [k] and the additional conditions (Type 1
and Type 2) described below. Here [C; € C>s] denotes the Iverson bracket.

Type 1 Condition: For each i € [k] we add the condition given in Equation 1. This is
called type 1 condition. Type 1 conditions ensure that the vertices in C; for all i € [k] are
protected. Define

IC = the collection of all clique type classes.

A vertex v from Cj is protected if and only if dg(v) > %, that is,

(@ = D[C; € K]+ D> 1x[C; € Ng(C)NCi]+ Y x;(C; € N (Ci) N Cso]
JElk] JElk]

> delv) ~ 1 (6.1)

2
Type 2 Conditions: Let x = (z1,...,xx) be the vector corresponds to S C V(G). We
want to make sure that the vector x = (xy,...,zx) or S forms a globally minimal defensive
alliance, that is, no proper subset of S forms a defensive alliance. Type 2 conditions ensure
that no proper subset of S is a defensive alliance. To do this, we use the idea of Proposition
5 in [12] which is a polynomial time algorithm to check whether a given defensive alliance
is globally minimal or not. Note that given a defensive alliance S, we will first remove an
arbitrary vertex v from it. If S is globally minimal, clearly S\ {v} cannot be a defensive
alliance. To get a defensive alliance we must delete all the unprotected vertices of S\ {v}. In
this way, if we keep on removing all the unprotected vertices then at the end we must be left

with an empty set otherwise the non-empty set remaining at the end will form a defensive
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alliance which is impossible if S is a globally minimal defensive alliance. We repeat this for
all vertices v in S. Note that the vertices in a type class have the same number of defenders
in S and the same number of attackers outside S. This implies if one vertex of a type
class becomes unprotected then all the vertices of that type class become unprotected. As
the neighbourhood diversity of GG is bounded by k, we guess an ordering of type classes in
which we want them to become unprotected. We now summarize the above discussion in

the following lemma.

Lemma 6.1.3. Let [y Ulso = {iy,la,...,4,}, 7 < k. Then, S is a globally minimal defensive

alliance in G if and only if for every i; € I; U I>5 there exists

a permutation of elements of I; U I, it 4 € I>9

a permutation of elements of (I \ {i;}) U Ise, ifi, € 4

such that

1. Vertices in Ci(;,y are unprotected in S\ {v} for any v € Cj,.

2. Define Siiy ;) = (S \ {v}) \ Crayy and Sray = Spaq,_,y \ Crag,y for all j > 1. Then,
the vertices in Ci,, ) are unprotected in Sz ) for all j > 1 and finally S ;) = 0

if 4, € IZQ, otherwise Sﬂil (ip_1) = 0 if 1 € 1.

1

Proof. The forward direction follows from the idea of Proposition 5 in [12]. Suppose S is
globally minimal in G. Clearly S\ {v} cannot be a defensive alliance for any v € S; suppose
v is in the type class C;,. To get a defensive alliance we must delete all the unprotected
vertices of S\ {v}. The unprotected vertices of S\ {v} are in some type class. Suppose
the unprotected vertices are in the type class Cli ;). Note that if one vertex of Cri ;) is
unprotected in S\ {v} then all the vertices of CLi ;) are unprotected in S\ {v}. Thus we
remove all the elements of C ;) from S\ {v} and set S (;,) = (S\ {v}) \ Cri(yyy- Clearly,
Spir(;yy cannot be a defensive alliance in G as S is a globally minimal defensive alliance.
Suppose the unprotected vertices of Si(;,) are in the type class Cri(;,). Thus we remove all
the elements of Ci ;) from S i ;) and set S ;) = i) \ Criny)- Again, using the same
argument, S (;,y cannot be a defensive alliance in G, so remove all the unprotected elements
of Sri(;,) and so on. In this way, if we keep on removing all the unprotected vertices then

at the end we must be left with an empty set otherwise the non-empty set remaining at the
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end will form a defensive alliance which is impossible as S is a globally minimal defensive

alliance.

The reverse direction follows from the definition of a globally minimal defensive alliance.

]

Definition 6.1.1. Given i; and 7%, we say S C V(G) is reducible by (i, 7%) if S satisfies

the conditions of Lemma [6.1.3

For given 7; and 7%, we want S to satisfy the conditions in inequality 2 and 3. These are
called type 2 conditions. By Lemma the vertices in Cri ;) must be unprotected in
S\ {v} for any v € Cy. Let u € CLi;,). Then u is unprotected in S\ {v} if and only if the

number of neighbours of u in S\ {v} is strictly less than %, that is,
(25, — D[Ci, € Nu(Criugiyy) NCo2] + > 1% [Cy € Nu(Crangiyy) NCllj # 1)+
Jelk]

Z 2j[Cy € Nu(Crivgyy) NCxal[j # U] + (03y) — D[Crivyy €K
JE[K]

| < de(u) —1 (6.2)

Let i; be the jth element of I; U Iy and P(i;) = {7 (i1), 7" (i2), ..., 7 (i;)} for j > 1. Let
u € Chiy,,,)- Then u is unprotected in S ;) if and only if the number of neighbours of u

in Sri(;,) 1s strictly less than %, that is,

(i, = Dlis ¢ P(i)][Cy, € N (Cringyy, ) N Coal+
Z 1 x [Cz € NH(Oﬂil(ij+1)) ﬂclHZ ¢ p (Z]) U {”}]—’_

1€[k]
> " wi[Ci € Nu(Crungyy,yy) N Coalli ¢ PP (i) U {ir}]
i€[k]
da(u) —1
T @gnges — DICoign € K < % (6.3)

Our algorithm for GMDA will use the following annotated problem as subroutine. In the
ANNOTATED GMDA problem, we are given a graph G, type classes C1, (s, ..., Cy of G, two

disjoint subsets Iy, Is5 of {1,2,...,k}, a permutation 7% for each 4, € I} U Is5 where

a permutation of elements of I; U I>, it 4, € Iso

a permutation of elements of (I \ {i;}) U Ise, ifd, € L4
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and the goal is to find a largest set S C V(G) such that SNC; # 0 if i € [; U s and S is
reducible by (i;, ) for all i; € I} U I>.

An algorithm for ANNOTATED GMDA: Let (G, (Ch,...,Cy), I1, Is2, ™ Vi, € [[UIsy) be
an instance of ANNOTATED GMDA. We reduce the problem of solving ANNOTATED GMDA

to an integer linear programming optimization with at most k£ variables as follows:

Maximize |I1’ + Z Z; [CZ S 622]

ic(k]
Subject to
v, =1V 1€l
2< ;< \C-|:n,- i € Iso
Equation (1
Equation (2
Vel Vje[r—1]

) v

) Y 'LlEIlU]>2

)

) vu€[>2 VJE[]

(
Equation (3
Equation (3

6.1.2 Running time

In our formulation for ANNOTATED GMDA, we have at most k& variables. The value of
the objective function is bounded by n and the value of any variable in the integer linear
programming is also bounded by n. The constraints can be represented using O(k?logn)
bits. Therefore, Lemmaimplies that we can solve the problem in k°®) . n®W time.

Lemma 6.1.4. If there exists an FPT algorithm for ANNOTATED GMDA then there exists
an FPT algorithm for GMDA.

Proof. Suppose there exists an FPT algorithm for ANNOTATED GMDA parameterized by
neighbourhood diversity k. Note that there are at most 3*(k!)* candidates for ANNOTATED
GMDA instances. The reason is this. There are at most 3* candidates for I; U I~ as each

C; has three options: either in Iy, I; or Isy; there are at most (k!)* candidates for n! for
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all [ € I U Is9. In order to obtain a largest globally minimal defensive alliance, we first
solve all ANNOTATED GMDA instances, then consider a largest solution over all ANNO-
TATED GMDA instances. Therefore, GMDA can be solved in FPT time parameterized by
neighbourhood diversity k. O

We have already proved that the ILP formula for an ANNOTATED GMDA can be solved
in k) . nO0) time. As there are at most 3*(k!)* many instances of ANNOTATED GMDA,
we can solve the GMDA in k**) . n°U time. Thus Theorem holds.

6.2 No polynomial kernel parameterized by vertex cover

number

A set C' C V is a vertex cover of G = (V, E) if each edge e € E has at least one endpoint
in X. The minimum size of a vertex cover in G is the vertex cover number of G, denoted
by ve(G). The problem is FPT parameterized by neighbourhood diversity implies that it
is FPT parameterized by vertex cover number ve. In this section we prove the following

kernelization hardness of GLOBALLY MINIMAL DEFENSIVE ALLIANCE.

Theorem 6.2.1. GLOBALLY MINIMAL DEFENSIVE ALLIANCE parameterized by the vertex

cover number of the input graph does not admit a polynomial compression unless coNP C

NP /poly.

To prove Theorem we give a polynomial parameter transformation (PPT) from the
well-known RED BLUE DOMINATING SET problem (RBDS) to GLOBALLY MINIMAL DE-
FENSIVE ALLIANCE parameterized by vertex cover number. Recall that in RBDS we are
given a bipartite graph G = (T'U S, E') and an integer k, and we are asked whether there
exists a vertex set X C S of size at most k such that every vertex in 7" has at least one
neighbour in X. We also refer to the vertices of T as terminals and to the vertices of S as

sources or nonterminals. The following theorem is known:

Theorem 6.2.2. [55] RBDS parameterized by |T'| does not admit a polynomial compression
unless coNP C NP /poly.
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Figure 6.1: PPT from RBDS to GLOBALLY MINIMAL DEFENSIVE ALLIANCE

6.2.1 Proof of Theorem [6.2.1]

By Theorem RBDS parameterized by |T'| does not admit a polynomial compression
unless coNP C NP /poly. To prove Theorem we give a PPT from RBDS parameterized
by |T'| to GLOBALLY MINIMAL DEFENSIVE ALLIANCE parameterized by the vertex cover
number. Given an instance I = (G = (T'U S, E), k) of RBDS, we construct an instance
I' = (G' k') of GLOBALLY MINIMAL DEFENSIVE ALLIANCE as follows. See Fig. for an

illustration.

e We introduce two new vertices z and 2, a set V.2 of 4n vertices, a set V= of |S| — k +
4n + 1 vertices, and a set V7 of 4n + 1 vertices. Make x adjacent to every vertex of

VAU SUVE and make x' adjacent to every vertex of V.2 U VL.

e For every vertex u € T, we introduce two vertices u; and uy, a set V.2 of 4n vertices,
a set V. of 4n + 1 vertices, and a set V] of 4n vertices. Make u; adjacent to every
vertex of {u} UV UV

), and make u adjacent to every vertex of V2 U V.

e We introduce two new vertices a and b into G/, a set V= of |T| + > (ds(u) — 1) + 2
ueT

vertices, and a set V= of |T'|+1 vertices. Make a adjacent to every vertex of TUVY, and
make b adjacent to very vertex of TU V" U{z'}. Let dg(u), u € T, denote the number
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of neighbours of u in S. For each u € T, we add a set Vi = {uf, u3,...,uf )} of

ds(u) — 1 vertices and make a and u adjacent to every vertex of V%

e Finally, we add a set T" = T UT of vertices where |T}| = |T| + 1 and |T5)| = 2. We

make every vertex of V2 U |J V.2 adjacent to every vertex of 7. For every s € S,
ueT

we make s adjacent to dr(s) + 1 many arbitrary vertices of 7. For every t € T, we
introduce d + 2 vertices and make them adjacent to ¢ where d is the degree of ¢ until
this point of the construction; these vertices are not shown in the figure. Finally, we
set ' = (S| —k)+4n+2+ |T|(4n+3) + 1.

Now we claim that there exists a vertex set X C S in G of size at most k£ such that every
vertex in T" has at least one neighbour in X if and only if G’ has a globally minimal defensive
alliance of size at least k’. Suppose there is a vertex set X C S in G of size at most k such

that every vertex in 1" has at least one neighbour in X. We show that the set

H = (S\X) U{baxax/}u‘/zA U U({uau1?u2}UVuA) U U{ucllw"vugx(u)—l}

ueT ueT

is a globally minimal defensive alliance. Clearly |H| > k' and observe that every vertex in
H is marginally protected. Let u be an arbitrary element of H. If u is an element of T', then
Ny(u) = {bur} U{uf, ..., uf 1} U Ns\x(u) and Nye(u) = {b} U{uayy, -, tdgw-1} U
Nx(u). That is, dg(u) = ds(u) + 1 and dge(u) = dg(u) + 1; thus u is marginally protected.
Similarly, it is easy to check that other vertices of H are also marginally protected. We also

see that G'[H| is connected. Using Observation 2, H is a globally minimal defensive alliance.

To prove the reverse direction of the equivalence, we assume that there exists a globally
minimal defensive alliance H of size at least k’. By Observation H can never contain a
vertex of degree one. As one degree vertices cannot be part of the solution, we observe that
no vertex from 7" is part of the solution. This is true because we will not be able to protect
any vertex from T as more than half of the neighbours are one degree vertices. We claim
that T C H. For the sake of contradiction suppose that there exists some u € T' such that
u & H. Then vertices in V2 U {uy,us} cannot be inside H as a globally minimal defensive
alliance must be connected. If we do not include V> inside H then H cannot achieve the size
k'. Therefore, we must include v in H. From the above argument, we also see that we must
include u; and uy inside H as otherwise vertices in V.2 cannot be protected. Therefore, we
have |J (V2 U {u,uy,us}) C H. Similarly, we can argue that V2 U {x,2’,b} C H. Observe

ueT
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that u € T must be marginally protected in H as otherwise H \ (V2 U {u1,us}) forms a
defensive alliance, which is not possible. For u € T', we have N(u) = Ng(u) UV U {a,b,us}
and hence d(u) = 2dg(u) + 2. Since a ¢ H and b,u; € H, we must have added at most
ds(u) — 1 nodes from Ng(u) in H for each uw € T. Consider X = H°N S. Clearly, every
vertex u € T has at least one neighbour in X. Next, we see that |X| < k. As otherwise,

|ISNH| < |S|—k and then x cannot be protected. This implies that I is a yes instance. [

6.3 Hardness Results

In this section, we show that GLOBALLY MINIMAL DEFENSIVE ALLIANCE is W][1]-hard
when parameterized by the size of a vertex deletion set into trees of height at most three,
i.e., a subset R of the vertices of the graph such that every component in the graph, after
removing R, is a tree of height at most three. We give a reduction from MULTI-COLORED
CLIQUE. The input of MULTI-COLORED CLIQUE consists of a graph G, an integer k, and a
partition (Vi, ..., V}) of the vertices of Gi; the task is to decide if there is a k-clique containing

exactly one vertex from each set V.

Theorem 6.3.1. GLOBALLY MINIMAL DEFENSIVE ALLIANCE is W|[1]-hard when parame-

terized by the size of a vertex deletion set into trees of height at most 3.

Proof. The approach for using MULTI-COLORED CLIQUE in reductions is described in [46],
and has been proven to be very useful in showing hardness results in the parameterized
complexity setting. We use G to denote a graph colored with k colors given in an instance of
MurLTI-COLORED CLIQUE, and G’ to denote the graph in the reduced instance of GLOBALLY
MINIMAL DEFENSIVE ALLIANCE. For a color i € [k], let V; denote the subset of vertices in
G colored with color ¢ and for a pair of distinct colors 7, j € [k], let E;; denote the subset of

edges in GG with endpoints colored i and j.

We construct G using two types of gadgets. Our goal is to guarantee that any globally
minimal defensive alliance in G’ with a specific size encodes a multi-colored clique in G.
These gadgets are the selection and validation gadgets. The selection gadgets encode the
selection of k vertices and (’;) edges that together encode a vertex and edge set of some multi-
colored clique in G. The selection gadgets also ensure that in fact k distinct vertices are

chosen from k distinct color classes, and that distinct (g) edges are chosen from (S) distinct
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edge color classes. The validation gadgets validate the selection done in the selection gadgets
in the sense that they make sure that the edges chosen are in fact incident to the selected
vertices. In the following we sketch the construction of selection and validation gadgets as

given in [14]:

Selection: For each color-class i € [k], and each pair of distinct colors i, j € [k], we construct
an i-selection gadget and an {i, j}-selection gadget which respectively encode the selection
of a vertex colored i and an edge colored {i,j} in G. The i-selection gadget consists of
a vertex x, for every vertex v € V; , and likewise, the {i, j}-selection gadget consists of a
vertex xy, ) for every edge {u,v} € Ey; j3. There are no edges between the vertices of the

selection gadgets, that is, the union of all vertices in these gadgets is an independent set in G'.

Validation: We assign to every vertex v in G two unique identification numbers, low(v) and
high(v), with low(v) € [n] and high(v) = 2n — low(v) where n is the order of the graph G.
For every pair of distinct colors i, j € [k], we construct validation gadgets between the {i, j}-
selection gadget and the i- and j-selection gadget. Let ¢ and j be any pair of distinct colors.
We describe the validation gadget between the i- and {i, j}-selection gadgets. It consists of
two validation vertices cy; and f;;, the validation-pair of this gadget. The first vertex a;; of
this pair is adjacent to each vertex z,,u € V;, by high(u) parallel edges, and to each edge-
selection vertex xyy ., {u, v} € Eyjy and v € V; , by low(u) parallel edges. The other vertex
f; is adjacent to each w,,u € V;, by low(u) parallel edges, and to each x, .y, {u,v} € Ey
and v € V}, by high(u) parallel edges. We next subdivide the edges between the selection
and validation gadgets to obtain a simple graph, where all new vertices introduced by the
subdivision are referred to as the connection vertices. The set of connection vertices adjacent
to one of the validation vertices {a;;, 5;;} and z,, is denoted by A}‘j and the set of connection

vertices adjacent to one of the validation vertices {cj, 8;;} and (. is denoted by Bjf.

For each connection vertex we add two new vertices and make them adjacent to it. For
each x, in the i-selection gadget, we introduce d(x,) new vertices and make them adjacent to
z,, and likewise for each xy, .} in {7, j}-selection gadget we introduce d(x(,.}) new vertices
and make them adjacent to xy,.). Let L be the set of all validation vertices in G'. Set
N = 100n2.

For every validation vertex a € L, we add the following protection gadget. We introduce
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Figure 6.2: A graphical depiction of the validation gadget. In the example, n = 5 and low(u)
= 3. Note that A}; contains the connection vertices in the yellow region and B} contains the
connection vertices in the pink region. The red vertices are one-degree vertices, and hence
they are not part of any globally minimal defensive alliance.

a new vertex a®

, a set V2 of N vertices, and a set of VaDA of N vertices. We make «
adjacent to every vertex of V.2; make a” adjacent to every vertex of V2 U VQDA. For each

vertex x € V2, we introduce two new vertices and make them adjacent to x.

Finally for every validation vertex oo € L, we add the following marginal protection gadget.
This gadget ensures that « is marginally protected in a globally minimal defensive alliance.
We introduce two new vertices o and o/®, a set Vof of N vertices, a set V7 of N vertices,
and a set V5, of N + 1 vertices. We make o adjacent to every vertex of {a} U vouvy
make o/® adjacent to every vertex of Vﬁ U VaD,A. For each vertex x € Vﬁ, we introduce two
new vertices and make them adjacent to x. Let d be the degree of o up to this point of
construction. Finally for each o € L, we introduce a set V2 of N +4n — d + 1 vertices and
make them adjacent to «. This completes the construction of graph G'.

We set k' =k + (g) + Sn(g) +2(4N +38) (g) We observe that on removing
R = {aij,aji,aﬁ,aﬁ,a;j,a;i,a;f,a;f c i, je k]l i#g}

U {Bis, By, By B By, Bias B B+ i g € (k)i # 5}

from graph G’, we are left with trees of height at most 3. Note that |R| = 16(];), a function

of k only. We claim that G has a k-clique with exactly one vertex from each V; if and only

138



Figure 6.3: A graphical depiction of protection and marginal protection gadgets associated
with validation vertex a. Note that the red vertices are one-degree vertices, and hence they
are not part of any globally minimal defensive alliance.

if G’ has a globally minimal defensive alliance of size at least k. Suppose that v; € Vi,

vy € Vo,..., v, € Vi is a k-clique in G. We show that

S={xy i€k} U{rpuy t LjEkli£jU |J ANUBIYUAZUBY"

i,j€[k],i#]
A A A A
U | Aag.of, a0 85,85, 8. 85 U VE UV, UV UV
i,j€[k],i#]
A A A A
U U {Oéj“&]l?aj’z?ajz 76]17/8‘]7,’6‘717 } UV UV UV UVﬁ;l
i,j€[k],i#7

is a globally minimal defensive alliance. Clearly |S| = k. To prove that S is a globally
minimal defensive alliance, we prove that S is connected and every vertex in S is marginally

protected. It is easy to see that each vertex in

{zy, 1€ K]} U{zpuny © i,j€[K],i#]}

is marginally protected as all the connection vertices adjacent to it are inside the solution

and the same number of one degree neighbours are outside the solution. It is also easy to see
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that every connection vertex in  |J A} UB;;"” U AJ UB;/" is marginally protected as
i,J€[k]i#]
it has two neighbours inside the solution and two neighbours outside the solution. Similarly,

we observe that all the vertices in the set

A
U {aij7a;]7 13’6”751]75 }U a”UV UVﬁ UVB;J

i,j€[k] 7]
A /A A A A A
U | A{egidl ot 85,885 UVE U Var UV UV
ijElk] it

are also marginally protected. Lastly, we prove that the vertices in theset |  {ayj, 5ij, i, Bji}
i,J€[K]i#]
are marginally protected. Consider «;j; it has total 2n connection vertices neighbours inside

the solution as high(u) + low(u) = 2n. Since Va%j C S and VaDZ,j NS = 0, note that, including
itself, a;; has N+2n+1 neighbours in S and dge(a;;) = (d—2n)+(N+4n—d+2) = N+2n+1.
Therefore o;; is marginally protected. It is easy to observe that S is connected. This shows

that S is a globally minimal defensive alliance.

In the reverse direction, we assume that G’ admits a globally minimal defensive alliance
S of size at least k’. By Observation 1, no vertex of degree one can be part of a globally
minimal defensive alliance of size > 2 as a one-degree vertex itself forms a defensive alliance.

We claim that the vertices in

A A A
U {am,ozw,ozw,aw,sz,ﬁm,ﬁw,ﬂ }UVAUV UVBZ]UV%

i,j€[k]i#]

g A
J U {aﬂ’a a; aﬂ’ﬁj“ﬁﬂ’ﬁﬂ’ ,‘i}UVinVaQiUVﬁ UV

Jio g0 B;z
i,j€[k],i#]

are always in S. Assume, for the sake of contradiction, that «;; ¢ S. Then we cannot
include any vertex from chj in the solution. This is true because every globally minimal
defensive alliance must be connected. If we cannot include the set Va%j then clearly |S| < ¥/,
a contradiction. Next, we observe that the protection of o;; requires at least one vertex
from the set Vafj inside the solution. As every vertex in Vafj has two one degree neighbours,
it implies that the protection of that vertex requires ozA inside the solution. Now, the
protection of a - forces the full set VA, inside the solution as its one-degree neighbours are
always outside S Similarly, we argue that «f; and V will be inside the solution. This

Qij
proves the claim.
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Observe that the above set has size exactly equal to 2(4N + 8)(’;) We need to add at least
k+ (g) +8n (S) more vertices in S. We claim that

U AjuBj"uAiuBi" CS.

1]
i,j€[k],i#£]

K
VO{A,H) forms a defensive alliance. This is equivalent to say that the marginal protection of o
ij
requires 2n neighbours from connection vertices inside the solution. Similarly, the marginal

Observe that a;; must be marginally protected inside the solution as otherwise S\ ({¢ a;f}u

protection of 3;; requires exactly 2n neighbours from connection vertices inside the solution.
Therefore, for each i,j € [k], i # j, we have A} U ijivj U A;Jj U B;fvi cS.

We now show that each vertex (or edge, respectively ) selection gadget contributes exactly
one vertex in S. We first show that every vertex selection gadget contributes at most one
vertex inside the solution. Suppose there exists a vertex selection gadget which contributes at
least two vertices inside the solution. Without loss of generality, suppose x,, and z,, from an
i-selection gadget are inside the solution. It implies that the protection of x,, and x,, requires
Aji and A7, respectively, inside the solution. Note that either ay; or f;; will have more than
2n connection vertex neighbours inside the solution as either high(u;) + high(us) > 2n or
low(u;) 4+ low(ug) > 2n. This is a contradiction as either a;; or f;; will not be marginally
protected inside S. We can argue similarly for edge selection gadgets and other color classes
as well. Next, we show that every vertex (or edge, respectively) selection gadget contributes
at least one vertex to the solution. For the sake of contradiction, assume that some i-selection
gadget does not contribute any vertex to the solution. In this case, it will not be possible to
protect the validation vertices a;; and 3;; for j # ¢, because no connection vertex between
i-selection gadget and the validation pair {o;, 3;;} can be added to the solution. This is
true as the protection of connection vertices require their neighbour in the ¢-selection gadget
to be part of the solution. As the edge selection gadget can contribute at most one vertex
due to above argument, the vertices in the set {a;;, 5;;} will have < 2n neighbours from
the set of connection vertices. This makes the protection of the validation vertices «;; and
Bi; impossible. Therefore each selection gadget contributes exactly one vertex inside the
solution.

Next, we claim that if the i-selection gadget contributes z,, and if the j-selection gadget

contributes x,, then the {7, j}-selection gadget must contribute xy,, ,,3. Assume, for the
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sake of contradiction, that the {i,j}-selection gadget contributes wy,, ;3 where vy # v;.
In this case, we have A}’ U AjJ U B"* U B;]™ C S. Note that one of the vertices from
the set {w;, 8;;} is not protected because when v; # vy either high(v;)-+low(vy) < 2n or
low(v;)+high(vg) < 2n. This is a contradiction. Therefore, we proved that if the i-selection
gadget contributes z,, and if the j-selection gadget contributes z,,, then the {i, j}-selection
gadget must contribute xy,, ,,3. It implies that the set {v; € G | x,, € i-selection gadget,i €

[k]} forms a multicolored clique in G. O

Clearly trees of height at most three are trivially acyclic. Moreover, it is easy to verify
that such trees have pathwidth [93] and treedepth [110] at most three, which implies:

Theorem 6.3.2. GLOBALLY MINIMAL DEFENSIVE ALLIANCE is W|[1]-hard when parame-

terized by any of the following parameters:

e the feedback vertex set number,
e the pathwidth and hence also treewidth of the input graph,

e the treedepth of the input graph.

6.4 NP-completeness

Rooted Globally Minimal Defensive Alliance asks for a globally minimal defensive alliance
S that contains a specified vertex r in a graph GG. The vertex r is called the root of S. A
globally minimal defensive alliance of G' will not be of use for this problem unless the set

contains r. We define the problem as follows:

ROOTED GLOBALLY MINIMAL DEFENSIVE ALLIANCE
Input: An undirected graph G = (V, E), a vertex r € V.

Question: Does there exist a globally minimal defensive alliance S C V/, such that r € S?

In this section, we prove the following theorem:
Theorem 6.4.1. ROOTED GLOBALLY MINIMAL DEFENSIVE ALLIANCE is NP-hard.
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Proof. We prove it is NP-hard by giving a polynomial time reduction from CLIQUE on regu-
lar graphs to ROOTED GLOBALLY MINIMAL DEFENSIVE ALLIANCE. See Figure for an
illustration. Let I = (G, k) be an instance of CLIQUE, where G is an s-regular graph. We
construct an instance I’ = (G',r) of ROOTED GLOBALLY MINIMAL DEFENSIVE ALLIANCE

as follows. First, we introduce one vertex r and a set of vertices {z1, 21, ..., 2, 2} into G'.

Figure 6.4: Reduction from CLIQUE to ROOTED GLOBALLY MINIMAL DEFENSIVE AL-
LIANCE

We make the set {21, 21,..., 2,2} & clique K in G’. We make every vertex of K adjacent
to every vertex u of G and the vertex r. Next, we introduce a set V7 of n — 2k vertices and
make them adjacent to r. For every u € V(G), we introduce a set V™' of n—s—2 vertices and
make them adjacent to u. This completes the construction of G’. To prove the correctness
of the reduction, we claim that G has a k-clique if and only if G’ admits a globally minimal
defensive alliance containing r. Assume first that G has a clique C of size k. We claim that
S = C UK U{r} is a globally minimal defensive alliance of G'. We observe that all the
vertices in S are marginally protected and G[S] is connected. Using Observation S is

a globally minimal defensive alliance containing r in G.

To prove the reverse direction of the equivalence, suppose G’ has a globally minimal
defensive alliance S containing vertex r. By Observation [I.3.1] one degree vertices cannot
be part of S. This implies that the protection of r requires all the vertices of K in the
solution. Therefore, we can assume that K C S. We observe that every vertex of K needs
at least k vertices from V(@) for its protection. We also see that, if we take more than k

vertices from V(@) inside S then all the vertices in K are overprotected. Then S\ {r} is also
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a defensive alliance. This is a contradiction to the assumption that S is globally minimal
defensive alliance. This proves that V(G) contributes exactly k vertices in the solution. Let
us denote this set by C. Since G is s-regular, note that each u € C requires exactly &k — 1
neighbours from C' for its protection. Then u has n — 2k + k —1 =n — k — 1 neighbours in
Sand (n —s—2)+ (s —k+1) =n —k — 1 neighbours outside S. This implies that C is a

clique of size exactly k. m

6.5 Closing Remarks and Future Directions

The main contributions in this work are that GLOBALLY MINIMAL DEFENSIVE ALLIANCE
is FPT when parameterized by neighborhood diversity; no polynomial kernel parameterized
by vertex cover number; and the problem is W[1]-hard parameterized by a wide range of
fairly restrictive structural parameters such as the feedback vertex set number, pathwidth,
treewidth, and treedepth of the input graph. We also proved that given a vertex v € V(G),
deciding if G has a globally minimal defensive alliance containing v, is NP-complete. The
parameterized complexity of GLOBALLY MINIMAL DEFENSIVE ALLIANCE remains unsettled
when parameterized by the solution size. It would be interesting to consider the parameter-
ized complexity with respect to structural parameters twin-cover and vertex integrity. The
modular width parameter also appears to be a natural parameter to consider here; since
there are graphs with bounded modular-width and unbounded neighborhood diversity, we

believe this is also an interesting open problem.
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Chapter 7

Offensive Alliances in Graphs

7.1 Introduction

The OFFENSIVE ALLIANCE problem has been studied extensively during the last twenty
years. A set S C V of vertices is an offensive alliance in an undirected graph G = (V, E) if
each v € N(S) has at least as many neighbours in S as it has neighbours (including itself)
not in .S. We study the classical and parameterized complexity of the OFFENSIVE ALLIANCE
problem, where the aim is to find a minimum size offensive alliance. We enhance our under-
standing of the problem from the viewpoint of parameterized complexity by showing that
(1) the problem is W|[1|-hard parameterized by a wide range of fairly restrictive structural
parameters such as the feedback vertex set number, treewidth, pathwidth, and treedepth of
the input graph; this puts it among the few problems that are FPT when parameterized by
solution size but not when parameterized by treewidth (unless FPT=W]|1]), (2) the problem
cannot be solved in time O*(2°*1°¢k)) where k is the solution size, unless ETH fails, (3) it
does not admit a polynomial kernel parameterized by solution size and vertex cover of the
input graph. On the positive side we prove that (4) it can be solved in time O*(vc(G)?®))
where vc(G) is the vertex cover number of the input graph. (5) it admits an FPT algorithm
when parameterized by vertex integrity of input graph. In terms of classical complexity, we
prove that (6) the problem cannot be solved in time 2°(") even when restricted to bipartite
graphs, unless ETH fails, (7) it cannot be solved in time 20(v") even when restricted to apex

graphs, unless ETH fails. We also prove that (8) it is NP-complete even when restricted to
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bipartite, chordal, split and circle graphs. This chapter is based on the papers [67] [60].

7.2 W|[1]-Hardness Parameterized by Structural Param-

eters

In this section, we show that OFFENSIVE ALLIANCE is W/[l]-hard parameterized by the
size of a vertex deletion set into trees of height at most seven, that is, a subset D of the
vertices of the graph such that every component in the graph, after removing D, is a tree of
height at most seven. On the way towards this result, we provide hardness results for several

interesting versions of OFFENSIVE ALLIANCE which we require in our proofs.

The OFFENSIVE ALLIANCEF

problem generalizes OFFENSIVE ALLIANCE where some ver-
tices are forced to be outside the solution; these vertices are called forbidden vertices. This

variant can be formalized as follows:

F

OFFENSIVE ALLIANCE
Input: An undirected graph G = (V, E), an integer r and a set V5 C V of forbidden
vertices such that each degree one forbidden vertex is adjacent to another forbidden vertex

and each forbidden vertex of degree greater than one is adjacent to a degree one forbidden

vertex.
Question: Is there an offensive alliance S C V such that (i) 1 < |S] < r, and (i)
SNVp =07

The STRONG OFFENSIVE ALLIANCEFN

problem is a generalization of STRONG OFFENSIVE
ALLIANCEY that, in addition, requires some “necessary” vertices to be in S. This variant

can be formalized as follows:

STRONG OFFENSIVE ALLIANCEFN

Input: An undirected graph G = (V| F), an integer r, a set Vo C V of necessary
vertices, and a set Vo C V of forbidden vertices such that each degree one forbidden
vertex is adjacent to another forbidden vertex and each forbidden vertex of degree greater
than one is adjacent to a degree one forbidden vertex.

Question: Is there a strong offensive alliance S C V such that (i) 1 < |S| < r, (ii)
SNVh=0,and (iii) Vo C S?
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While OFFENSIVE ALLIANCE asks for offensive alliance of size at most r, we also consider
ExAcT OFFENSIVE ALLIANCE that concerns offensive alliance of size exactly . Analogously,
we also define exact versions of STRONG OFFENSIVE ALLIANCE presented above. To prove
Lemmal(7.2.2] we consider the following problem:

MULTIDIMENSIONAL RELAXED SUBSET SUM (MRSS)

Input: Two integers k and &/, a set S = {s1,...,s,} of vectors with s; € N* for every i
with 1 < i < n and a target vector ¢t € N*.

Parameter: k + £’

Question: Is there a subset S” C S with |S’| < k' such that > s> t?
ses’

Lemma 7.2.1. [74] MRSS is W[1|-hard when parameterized by the combined parameter

k + k', even if all integers in the input are given in unary.

We now show that STRONG OFFENSIVE ALLIANCEF N is W]1]-hard parameterized by the

size of a vertex deletion set into trees of height at most 5, via a reduction from MRSS.

Lemma 7.2.2. STRONG OFFENSIVE ALLIANCEL N is W][1]-hard when parameterized by

the size of a vertex deletion set into trees of height at most 5.

Proof. To prove this we reduce from MRSS, which is known to be W[1]-hard when parameter-
ized by the combined parameter k+£’, even if all integers in the input are given in unary |[74].
Let I = (k, K, S,t) be an instance of MRSS. We construct an instance I’ = (G, r, Va, Vp) of
STRONG OFFENSIVE ALLIANCEF N the following way. See Figure for an illustration.
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Figure 7.1: A schematic view of the construction of G in the proof of Lemma for a
MRSS instance S = {(2,1),(1,1),(1,2)}, t = (3,3), k = 2 and k' = 2. We introduce k = 2

new vertices {uy, us} in G. For uy, we introduce a set V,,,n of > s(1) = 2+1+41 = 4 forbidden
ses

vertices and a set Vi, o of > s(1)—2t(1)+2 = 2x4—2%x3+2 = 4 necessary vertices. Similarly
ses
we introduce four forbidden and four necessary vertices for uy. For the vector s; = (2,1), we

introduce a tree structure Ty, with vertex set Ay, UB,, UAS UBJ UC,, UZ,, U {szl s Ysys Zsy (-
As maX(Sl) =2, Ay, = {ailvaglaagl}v B, = {bil7b;17b§1}v Asljl = {a?81>a5817a§81}7 BSD1 -
{75 551 b2%1) have three vertices each; Cy, has 2max(s;) + 2 = 6 vertices. Similarly, for
the vectors so = (1,1) and s3 = (1,2), we create tree structures Ty, and T}, respectively.

The edges incident to a are depicted in pink colour.
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Let s = (s(1),s(2),...,s(k)) € S and let max(s) denote the value of the largest coordinate

of s. Set N = > (2max(s) + 2). We introduce three types of vertices: necessary vertices,
seS
forbidden vertices and normal vertices. We often indicate necessary vertices by means of

a triangular node shape, forbidden vertices by means of a square node shape, and normal
vertices by means of a circular node shape. We want to make sure that necessary vertices
are always inside every solution, forbidden vertices are always outside every solution, and
a normal vertex could be inside or outside the solution. The vertex set of the constructed

graph G is defined as follows:

1. We introduce a set of k necessary vertices U = {uq, us, ..., ux}. For every u; € U, we
create a set Vo of > s(i) forbidden vertices and a set V,, A of 2> s(i) — 2t(i) + 2
ses ses

necessary vertices.

2. For each vector s = (s(1),s(2),...,s(k)) € 5, we introduce a tree T into G. The

vertex set of tree T is defined as follows:
V(T,) = A,UuB,UAJUBYUC,UZ, U {xyz}

where Ay = {ai,.... a1t Bs = {01, 0hxiya b AD = {dPs, ... et (s)41)

> “max(s > “max
O _ Os Os _ s s 3
By = {b7", .. bpeyen s and Cs = {ci, ..., 65, (o)10) are five sets of vertices, and
As _Ns As As A : .
the set Z, = {27, 25", 25", 25", 25, 27} contains five necessary vertices and one

forbidden vertex.

3. We introduce a vertex a and a set of four vertices A = {a7, a5, a3 ,a"} containing

three necessary vertices and one forbidden vertex.

We now create the edge set of G.

1. For every u; € U, make u; adjacent to every vertex of V,, o U V,,ao. For each i €
{1,2,...,k} and for each s € S, we make u; adjacent to exactly s(i) many vertices of

Ag in arbitrary manner.
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2. We now create the edge set of T%.

max(s)+1
B(T,) = {(ws,2), (o} U | {08, (0, a0), (0, 09). (20,0 }
=1
5 2 max(s)+2

UU{<ZsaziAs>7('zsa2DS)}U U (ysacf)

i=1 i=1

3. Make a adjacent to every vertex of A. For each s € S, we make a adjacent to every
vertex of A, U B, U (.

We now define i
Va=A\{duJViaulJZ\ {7}
=1 seS
and

k
Vo =UU{a,a”}u|J Vo U | AT UBY U{z, 27}

i=1 seS

k
We set r = ) 2( > s(i) —t(i) + 1) + > 2(max(s) + 1) + 5n + 3+ k’. Observe that if we
=1 ses ses
remove the set U U {a} of k+ 1 vertices from G, each connected component of the resulting

graph is a tree with height at most 5. Note that I’ can be constructed in polynomial time.

It remains to show that I is a yes instance if and only if I’ is a yes instance. Towards

showing the forward direction, let S’ be a subset of S such that || < & and > s>t. We
seS’
claim

R=VaulJ (A uB,u{z})u | C.
ses’ seS\S’

is a strong offensive alliance of G such that |R| < r, VA C R, and V5N R = (). Observe that

NR)=UU{a}u | J (AJu{=})u |J {v}

ses’ seS\S!
Let u; € U, then we show that dg(u;) > dge(u;) + 2. The neighbours of u; in R are the
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vertices of V,, A and s(i) vertices of A, for each s € §'. As > s(i) — t(i) > 0, we get
ses’

dr(ui) =Y 5(0) + [Vua|

ses’
= s(i)+2) s(i) — 2t(i) + 2
ses’ ses

Vv
size of Vi,

= (D s — 1) + D2 s0) — 1) + > s(6) + 2

ses’ seS sES

J

~
>0 by assumption

> (i) — @) + > s(i) +2

seSs seS
= 7 s+ (Do) =) + D s0) +2
seS\S’ _ s€s | ses
>0 by a;;nnption
> > s(i)+ Y s(i) +2= > os(i)+ Vel 42
seS\S’ s€S s€S\ S’
\W—/ (. ~ /
size of VuiD the number of neighbours of u;in R¢
= dRc (U1> -+ 2.

For the remaining vertices z in N(R), it is easy to see that dg(z) > dge(x) 4+ 2. Therefore,

R is a strong offensive alliance.

Towards showing the reverse direction of the equivalence, suppose G has a strong offensive

k
alliance R of size at most r such that VA € R and Vo N R = 0. As |J Vi,a C Va, the
=1

%
k

vertices of |J Vi,a are in the solution R. On the other hand, as U C Vf, the vertices
i=1

of U are not in R. Therefore it may be noted that U C N(R). We know VA contains

k

> ( > 2s(1) — 2t(z’)> + 5n + 3 vertices; thus besides the vertices of Va, there are at most

i=1 ‘seS

ZS 2(max(s)+ 1)+ & vertices in R. As the necessary vertices a’', a5, ag are in R and a € Vj

s€

is not in R, we have a € N(R). Note that N(a) = {a{, a5, a5, a"} |J (A, U B, U C,) and
ses

dg(a) = Y 4(max(s) + 1) +4. As a is adjacent to three necessary vertices, it must have at
ses
least ) 2(max(s)+1) many neighbours in R from the set |J (A;UBsUCs). It is to be noted
seS seS
that if a vertex from the set A; U By is in the solution then the whole set Ay U By U {z4}
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lie in the solution. Otherwise v € AY C N(R) will have dg(v) < dge(v) + 2 which is a
contradiction as R is a strong offensive alliance. This shows that at most & many sets of the
form As U Bs U {z4} contribute to the solution as otherwise the size of solution exceeds 7.
Therefore, any strong offensive alliance R of size at most r can be transformed to another

strong offensive alliance Ry of size at most r as follows:

Ry=Vau (AsuBsu{xs}>u U o

zs€ER 2sEV(G)\R

We define a subset S = {s €S|z € RO}. Clearly, |S'| < k’. We claim that > s(i) > t(i)
sesS’
for all 1 < ¢ < k. Assume for the sake of contradiction that ) s(i) < (i) for some
ses’

i€{1,2,...,k}. Then, we have

dro(ui) = Y s(i) + [Vigal = Y s(i) +2)  s(i) — 2t(i) + 2

ses’! seS’! seS

J/

TV
size of Vi, n

= s(i) —t(i)+ Y s(i) — () + > s(i) +2

ses’ seS seS

<0 by assumption

<Y s(i) = t(i) + Y s(i) + 2

ses ses
= > s()+ (Z s(i) — t(z’)) +> (i) +2
seS\S’ s€s’ J ses

<0 by assumption

< E s(i) + E s(i) +2 = E s(t) + Vol + 2
seS\S’ seS seS\S’
——
size of Vu,0

= dRS (Ul) + 2
and we also know u; € N(Rp), which is a contradiction to the fact that Ry is a strong

offensive alliance. This shows that [ is a yes instance. O]

We have the following corollaries from Lemma m

FN

Corollary 7.2.3. STRONG OFFENSIVE ALLIANCE" - is W[1|-hard when parameterized by

the size of a vertex deletion set into trees of height at most 5, even when |Va| = 1.
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Proof. Given an instance I = (G, r,Va, V) of STRONG OFFENSIVE ALLIANCEFN, we con-

struct an equivalent instance I’ = (G',r", VL, V) with |V{| = 1. See Figure for an

illustration.

Figure 7.2: An illustration of the gadget used in the proof of Corollary

The construction of G’ starts with G’ := G and then add the following new vertices and
edges. Let vy, vq,...,v, be vertices of VA where we assume that ¢ > 1. We introduce two
vertices x and y where x is a forbidden vertex and y is a necessary vertex; and make x and
y adjacent. We make x adjacent to all the vertices in VA. We also introduce a set V5 of
¢ — 1 forbidden vertices and make them adjacent to z. Set ' = r + 1. Define Vi = {y} and
V4 = {x} UV,nU VG, We define G’ as follows

V(G) =V(G)U{z,y} UV,q

and

B(@) = B(G) U{(.y). (x.0).(2.5) | o € Vo, B € Va .

Let H be a vertex deletion set of G into trees of height at most 5. Clearly, if H has at most
k vertices then the set H U {z} has at most k + 1 vertices and it is a vertex deletion set of

G' into trees of height at most 5. We now claim that [ and I’ are equivalent instances.

Suppose GG has a strong offensive alliance R of size at most r such that Vo C R and
VaN R =10. Clearly R = RU {y} is a strong offensive alliance of size at most r + 1 in G’
such that VA C R’ and VN R’ = (. Note that € Ng/(R') and it satisfies dp/(x) =1+ 1 >
dpe(2) +2 = (1 — 1) + 2.

Conversely suppose G’ has a strong offensive alliance R’ of size at most r + 1 such that
y € R and VN R = 0. We claim {vy,vs,...,v} C R'. As y is in R’ but its neighbour x is
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not in R’, we have x € N(R') and it satisfies the condition dp/(x) > dge(x) 4+ 2. Note that
N(z) = {vi,ve, ..., 0} U{y} UV and Nge(z) = V. As x has [ — 1 neighbours outside
R’ and x satisfies the condition dgr/(z) > dge(z) +2 = (I —1)+2 = [+ 1, R includes
{v1,v9,...,v} U{y}. Define R = R'\ {y}. Clearly R is a strong offensive alliance of size at
most 7 such that Vo = {vy,ve,..., v} C Rand RNV = 0. O

We can get an analogous result for the exact variant.

FN

Corollary 7.2.4. EXACT STRONG OFFENSIVE ALLIANCE' *" is W[1]-hard when parame-

terized by the size of a vertex deletion set into trees of height at most 5 even when |Va| = 1.

Next, we present an FPT reduction that eliminates necessary vertices.

F

Lemma 7.2.5. OFFENSIVE ALLIANCE" is W][l|-hard when parameterized by the size of a

vertex deletion set into trees of height at most 5.

Proof. To prove this we reduce from the STRONG OFFENSIVE Arviancel' N

problem, which
is W[1]-hard when parameterized by the size of a vertex deletion set into trees of height at
most 5, even when |Vo| = 1. See Corollary Given an instance I = (G,r,Vao =
{z}, V) of STRONG OFFENSIVE ArLiancelf' N
of OFFENSIVE ALLIANCE the following way. See Figure for an illustration. The

construction of G’ starts with G’ := G and then add some new vertices and edges. Let

, we construct an instance I’ = (G',r', V{)

Figure 7.3: The reduction from STRONG OFFENSIVE ALLIANCEE N to OFFENSIVE

ALLIANCEY in Lemma [7.2.5] Note that the set {v1,...,vy} may contain forbidden ver-
tices of degree greater than one.

n be the number of vertices in G. Suppose V(G) = {z,v1,v2, ..., 0, f1,..., far} where
F ={fi,..., far} is the set of degree one forbidden vertices in V(G). Note that, we give

special consideration to degree one forbidden vertices fi,..., f,» in G. While constructing
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G' from G, we refrain from introducing any new neighbors for these vertices. In other words,
these vertices will retain their status as degree one forbidden vertices in G’ as well. This
is because, in each instance, we would like to maintain the property that each degree one
forbidden vertex is adjacent to another forbidden vertex and each forbidden vertex of degree
greater than one is adjacent to a degree one forbidden vertex. This structural property is
exploited in the proof of Theorem [8.2.1]to get rid of forbidden vertices. We introduce two
forbidden vertices t, 2" into G'. We create a set V" of 4n forbidden vertices and a set V"
of n forbidden vertices into G’. Finally we create a set T' = {t1,...,t4,} of 4n vertices into
G'. Make t” adjacent to every vertex of TU VP U {x} and make 2" adjacent to every vertex
of TUVEUV(G)\ F. Set r' = r + 4n. We define G’ as follows:

V(G =V uTuVSuvau{t? 2"}
and

E(G") =E(G) U {(tD,a) L aeTUVPU {x}}
U{(xm,ﬁ) : BGTUVEUV(G)\F}

We define V, = Vg U VE U VP U {t7, 27}, Observe that there exists a set of at most k + 2
vertices for G’ whose deletion makes the resulting graph a forest containing trees of height
at most 5. We can find such a set because there exists a vertex deletion set D of size at most
k for G into trees of height at most 5. We just add {z", "} to the set D, then the resulting
set is of size k + 2 whose deletion from G’ makes the resulting graph a forest containing trees
of height at most 5.

We now claim that [ is a yes instance if and only if I’ is a yes instance. Assume first that
R is a strong offensive alliance of size at most r in G such that {z} C Rand VoNR = 0. We
claim R’ = RUT is an offensive alliance of size at most r +4n in G’ such that VN R’ = (.
Clearly, N(R') = {t“,2”} U N(R). If v is an element of N(R), we know that dg(v) >
dge(v) + 2 in G. Therefore in graph G', we get dr/(v) > dge(v) + 1 for each v € N(R) due
to the vertex z5. If v € {z", 7}, then the neighbours of v that are also in R’ consist of the
4n elements of T' and the elements of R; thus dr/(v) > 4n + 1. The neighbours of x that
are outside R’ consist of the n elements of V.~ and the elements of V(G) \ (F U R); thus we

get dpe(2”) < 2n. The neighbours of ¢~ that are outside R’ consist of 4n elements of V;”
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only; thus dg«(t5) = 4n. Therefore dr/(v) > 4n + 1 > dge(v) + 1. This shows that I’ is a

yes instance.

To prove the reverse direction of the equivalence, suppose R’ is an offensive alliance of
size at most 7’ = r 4+ 4n in G’ such that ' NV, = 0. We claim that T U {z} C R'. Since
R’ is non empty, it must contain a vertex from the set TUV(G)\ F. Then z” € N(R') and
it satisfies the condition dgp:/(2”) > dgre(z”) + 1. Due to n forbidden vertices in the set V-,
node z” must have at least n + 1 neighbours in R’. This implies that R’ contains at least
one vertex from 7. Then t© € N(R') and it satisfies the condition dg/ (t9) > dge(t7) + 1.
Since |V,”| = 4n, the condition dg/(tY) > dpge(t”) + 1 forces the set {x} U T to be inside
the solution. Consider R = R'NV(G). Clearly |R| <r, x € R, RNV = 0 and we show
that R is a strong offensive alliance in G. For each v € N(R') N V(G) = N(R), we have
Ng/(v) > Npe(v) + 1 in G'. Notice that we do not have 7 in G which is adjacent to all
vertices in N(R). Thus for each v € N(R), we get Nr(v) > Nge(v) + 2 in G. Therefore R
is a strong offensive alliance of size at most r in G such that x € R and RNV = (). This

shows that [ is a yes instance. O

We have the following corollaries from Lemma

F

Corollary 7.2.6. EXACT OFFENSIVE ALLIANCE" is W][1]-hard when parameterized by the

size of a vertex deletion set into trees of height at most 5.

We are now ready to show our main hardness result for OFFENSIVE ALLIANCE using a

reduction from OFFENSIVE ALLIANCEF.

Theorem 7.2.7. OFFENSIVE ALLIANCE is W][1]-hard when parameterized by the size of a

vertex deletion set into trees of height at most 7.

Proof. We give a parameterized reduction from OFFENSIVE ALLIANCEY which is W|1]-hard
when parameterized by the size of a vertex deletion set into trees of height at most 5. See
Lemmal7.2.5 Let I = (G,r, Vq) be an instance of OFFENSIVE ALLIANCEY . Let n = V(G)|.
We construct an instance I’ = (G',7") of OFFENSIVE ALLIANCE the following way. We set

r" = r. Recall that each degree one forbidden vertex is adjacent to another forbidden vertex
and each forbidden vertex of degree greater than one is adjacent to a degree one forbidden
vertex. Let u be a degree one forbidden vertex in GG and u is adjacent to another forbidden

vertex v. For each degree one forbidden vertex u € Vg, we introduce a tree T, rooted at u
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of height 2 as shown in Figure The forbidden vertex v has additional neighbours from
the original graph G which are not shown in the figure. We define G’ as follows:

V(G) =V(G)U U V(T,)

and

E(G) = E(G)U U E(T,).

ueVg & dg(u)=1

Clearly G’ can be constructed in polynomial time. Let D be a vertex deletion set in G into

i
Figure 7.4: Our tree gadget T, for each degree one forbidden vertex u € Vg

trees of height at most 5. Each component of G’ — D is a tree with height at most 7. It
remains to show the equivalence between I and I’. It is easy to verify that if R is an offensive
alliance of size at most r in G such that R N Vo = (), then it is also an offensive alliance of

size at most ' = r in G".

To prove the reverse direction of the equivalence, suppose that G’ has an offensive alliance
R’ of size at most 7’ = r. We claim that no vertex from the set Vi |J V(7)) is part of R'.

ueVh
It is to be noted that if any vertex from the set Vi |J V(7)) is in R’ then the size of R’
ueVg
exceeds 2r. This implies that R = R’ N G is an offensive alliance such that RN V5 = () and
|R| < r. This shows that [ is a yes instance. O

We have the following consequences.

Corollary 7.2.8. EXACT OFFENSIVE ALLIANCE is W][1]-hard when parameterized by the

size of a vertex deletion set into trees of height at most 7.

Clearly trees of height at most seven are trivially acyclic. Moreover, it is easy to verify that
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such trees have pathwidth [93] and treedepth [110] at most seven, which implies:

Theorem 7.2.9. OFFENSIVE ALLIANCE and EXACT OFFENSIVE ALLIANCE are W|[1]-hard

when parameterized by any of the following parameters:
e the feedback vertex set number,

e the treewidth and pathwidth of the input graph,

e the treedepth of the input graph.

7.3 FPT Lower Bound Parameterized by Solution Size

We know that OFFENSIVE ALLIANCE admits an FPT algorithm when parameterized by the
solution size [50]. The algorithm in [50] uses branching technique and solves the problem in
O*(20k1gk)) time. We prove that this running time is essentially optimal assuming ETH.
Hardness for OFFENSIVE ALLIANCE follows from a reduction from k x k& (PERMUTATION)
HITTING SET WITH THIN SETS. In k£ x kK HITTING SET, we restrict the universe to a k x k
table. We define the problem as follows:

k x k HITTING SET

Input: A family F of sets Fy, Fy, ..., F,, C [k] x [k].

Question: Is there a set X C [k] x [k] containing exactly one element from each row
such that X N F; # () for any 1 <i < m?

In the £ x k (PERMUTATION) HITTING SET problem, we are given a family F of subsets
of [k] x [k], and we would like to find a set X, consisting of one element from each row and
induces a permutation of [k], such that X N F # () for each F' € F. In the thin set variant
we assume that each F' € F contains at most one element from each row. In the proof, we
will use the fact that & x & (PERMUTATION) HITTING SET WITH THIN SETS cannot be
solved in time 2°*1°ek) unless ETH fails [101].

Theorem 7.3.1. Unless ETH fails, OFFENSIVE ALLIANCE cannot be solved in time O* (20108 %))

where k s the solution size.

Proof. We provide a polynomial-time algorithm that takes an instance (F, k) of k x k (PER-
MUTATION) HITTING SET WITH THIN SETS, and outputs an equivalent instance (G,r) of
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OFFENSIVE ALLIANCE with » = 5k. We construct G the following way.

1. For every F' € F, we introduce a vertex vp into G. Let Ve = {vp | F € F}. We also
introduce a set of k? vertices W = {w;; : i € [k],j € [k]}. Make vp adjacent to w;; if
(i,j) € F.

2. We introduce a clique D® of size 4k into G. For every d € D*, we add a set of 10k
vertices and make them adjacent to d. For every F' € F, we make vy adjacent to every

vertex of D2,

3. We introduce another clique D" of size 12k + 1 into G. Let dp = dw (vr), the number
of neighbours of vr in W. As we are dealing with thin sets, we have dr < k for all

F € F. For every F € F, we make vp adjacent to any 4k — dp + 1 vertices of D".

4. For every row i € [k|, create a vertex r; into G and make r; adjacent to w;; for all
j € [k]. Let R={ry,...,m}. For every r € R, make r adjacent to every vertex of D*
and any 3k + 1 vertices of D".

5. For every column j € [k], create a vertex ¢; into G and make ¢; adjacent to w;; for all
i € [k]. Let C' = {ci,...,cx}. For every ¢ € C, make ¢ adjacent to every vertex of D>
and any 3k + 1 vertices of D".

This completes the construction of G. Set r = 5k. We now formally argue that instances
(F,k) and (G, r) are equivalent. Assume first that X is a solution to the instance (F, k).
For each i € [k], let j; be the unique index such that (7, j;) € X. We claim that the set

_ JAN
S— D U {wljl,ngQ,...,wkjk}

is an offensive alliance of size exactly 5k in G. We see that N(S) = RUCU{vr : F € F}.
Let v be an arbitrary element of N(S5). We need to prove that dg(v) > dge(v) + 1 for all
v € N(S). If v is an element of R or C, the neighbours of v in S are the elements of D®
and one element from W. Thus we have dg(v) = 4k + 1. The neighbours of v in S¢ are
3k + 1 elements of D" and k — 1 elements of W; therefore we have dgc(v) = 4k. If v is an
element of {vp : F € F}, the neighbours of v in S are the elements of D and at least
one element from W as X is a hitting set; thus we have dg(v) > 4k + 1. The neighbours of
vin S¢ are 4k — d + 1 elements of D" and at most d — 1 elements from W; thus we have
dse(v) < (4k —d+ 1) + (d — 1) = 4k. This shows that S is indeed an offensive alliance.
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Figure 7.5: Example of the reduction in Theorem applied to an instance (F =
{F1, F», F3},5) of 5 x 5 PERMUTATION HITTING SET WITH THIN SETS that has three sets

Fl - {<171)7(271)7(474)7(573)}7F2 - {(174)7(374>7(571)}7F3 - {(171)?(275)7<372)7(575)}

with each set containing at most one element from each row.

In the reverse direction, let S be an offensive alliance of size at most 5k in GG. First we
show that it can be assumed that N(S) N DY = (). Suppose, for the sake of contradiction,
that v € N(S)N DY. Then v must satisfy the condition dg(v) > ds-(v) + 1. As v has degree
at least 12k, in order to satisfy the condition dg(v) > dge(v)+1, the size of S must be at least
6k, a contradiction to the assumption that the size of S is at most 5k. Next we show that
it can be assumed that S N DY = (). Suppose that S contains an element of D”. As D" is a
clique and D"NN(S) = 0, if S contains one element of DY then DY C S. This is not possible
as D" has 12k + 1 elements and S has at most 5k elements. Therefore, we may assume that
(SUN(S))N D" = . This in turn implies that that (RUC UVZF)NS = 0. As the offensive
alliance S is non-empty, we have SN D* # () or SNW # (). Note that in either case, we get
N(S)N(RUCUVZ) # 0. Let u be an arbitrary element of N(S)N(RUCUVF). If SND> =
then clearly we have dg(u) < dgc(u) + 1 which is not possible. Therefore S N D> # (. We
observe that if S contains one element of D then it contains all elements of D®, that is,
DA CS. AsD?C Sand (RUCUVE)NS =0, we get (RUCUVz) C N(S). As S is an
offensive alliance, every element u of N(S) has to satisfy the condition dg(u) > dge(u) + 1.
Consider an arbitrary vertex r; of R. If SN {w;; : j € [k]} = 0 then dg(r;) = 4k and
dge(r;) = 4k which is not possible as r; does not satisfy the condition dg(r;) > dge(r;) + 1.
This implies that, for each ¢ € [k], S contains at least one element from {w;; : j € [k]}

but since |S| < 5k and D® C S, S contains exactly one element from {w;; : j € [k]}.
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Using the same argument for an arbitrary vertex c¢; € C, we get that S contains exactly
one element from {w;; : i € [k]}. We claim that X = {(4,7) | w;; € S} is a permutation
hitting set of size k. Let us assume that there exists a set F' € F which is not hit by X.
In that case, we have dg(vp) = 4k and dgc(vp) = (4k — dp) + dp = 4k. This means that
ds(vp) < dge(vp) + 1 which is a contradiction. Therefore, X is a hitting set of size k. As X

has exactly one element in each row and in each column, X is a permutation hitting set.

An algorithm solving OFFENSIVE ALLIANCE in time 2°F1°8%) would therefore translate
into an algorithm running in time 2°*1°¢%) for k x k (PERMUTATION) HITTING SET WITH
THIN SETS and contradicts the ETH. O

Corollary 7.3.2. Unless ETH fails, EXACT OFFENSIVE ALLIANCE problem cannot be

solved in time O*(2°F18 k) “where k is the solution size.

7.4 No polynomial kernel parameterized by solution size

and vertex cover

Parameterized by the solution size, the problem is FPT and in this section we show the

following kernelization hardness of OFFENSIVE ALLIANCE.

Theorem 7.4.1. OFFENSIVE ALLIANCE parameterized by the solution size and vertex cover

combined does not admit a polynomial compression unless coNP C NP /poly.

To prove Theorem we give a polynomial parameter transformation (PPT) from CLOS-
EST STRING to OFFENSIVE ALLIANCE parameterized by the solution size. In the CLOSEST
STRING problem we are given an alphabet X, a set of strings X = {x1,xs,..., 2} over ¥
such that |z;| = n and an integer d. The objective is to check whether there exists a string «
over ¥ such that dy(z,z;) <d, i € {1,...,k}, where dy(z,y) denotes the number of places
strings = and y differ at. Let = be a string over alphabet ¥. We denote the letter on the
p-th position of = as z[p]. Thus z = z[1]z[2]...z[n| for a string of length n. We say string
x and y differ on the p-th position if z[p| # y[p]. The following theorem is known:

Theorem 7.4.2. [8]/ CLOSEST STRING parameterized by the distance d and the length of
the strings n, does not admit a polynomial kernel unless NP C coNP /poly.
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They also observe that the kernelization lower bound for CLOSEST STRING works for any

fixed alphabet ¥ of size at least two. Therefore, without loss of generality, we assume that
Z - {Ab AQ}

7.4.1 Proof of Theorem |7.4.1

We give a PPT from the CLOSEST STRING problem. Given an instance (&X', d) of the CLOS-
EST STRING problem, we construct an instance (G, r) of OFFENSIVE ALLIANCE the following

way.

1. For every € X, we introduce a vertex v, into G. Let Vy = {v, | z € X}. We also
introduce a set of 2n vertices W = {w;; : i € [n],j € [2]}. Make v, adjacent to w;
if the letter on the ¢th position of x is A;; make v, adjacent to w;s if the letter on the

1th position of x is As.

2. We introduce a clique D® of size 3n + 2d + 1 into G. For every d € D*, we add a
set V; of 12n vertices and make them adjacent to d. For every v, € Vy, we make v,

adjacent to every vertex of D*.

3. We introduce another clique D" of size 12n + 1 into G. For every v, € Vr, we make

v, adjacent to any 4n vertices of DY,

4. For every row i € [n], create a vertex r; into G and make r; adjacent to w;; and w;s.
Let R = {ry,...,r,}. For every r € R, make r adjacent to any three vertices of D*

and any two vertices of D".

This completes the construction of G. Note that the set RUW U D" U D* forms a vertex
cover of G of size 18n + 2d + 2. We set r = 4n + 2d + 1. It is easy to see that the above
construction takes polynomial time. We now formally argue that instances (X, d) and (G, r)

are equivalent. Assume first that y is a solution to the instance (X, d), that is, dy(z;,y) < d
for all i € {1,2,...,k}. We claim that

S:DAU{wij\y[i]:Aj fori:1,2,...,n}

is an offensive alliance of size at most r. We see that N(S) = RU Vy. We need to prove
that dg(v) > dge(v) + 1 for every v € RU Vy. If r is an element of R, the neighbours of
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Figure 7.6: Example of reduction of Theorem applied to an instance (x, d) of CLOSEST
STRING where y contains three strings z; = 1011100, zo = 1101010, z3 = 1110001 and d = 3.
A solution string y = 1000000 is shown in red circles.

rin S are three elements of D> and one element from W. Thus we have dg(r) = 4. The
neighbours of 7 in S¢ are two elements of D" and one elements of W; therefore we have
dge(r) = 3 and r satisfies the required condition. If v, is an element of Vy, the neighbours
of v, in S are 3n + 2d + 1 elements of D and n — dy(x,y) element from W. Thus we have
ds(vy) > (3n+2d+ 1) + (n —d) = 4n + d + 1. The neighbours of v, in S¢ are 4n elements
of D¥ and dy(z,y) element from W; hence dse(v,) = 4n + dy(x,y) < 4n + d. Therefore v,

satisfies the required condition.

In the reverse direction, let S be an offensive alliance of size at most 4n + 2d + 1 in
G. First we show that it can be assumed that N(S) N DY = ). Suppose, for the sake of
contradiction, that v € N(S) N DY. Then v must satisfy the condition dg(v) > dge(v) + 1.
As v has degree at least 12k, in order to satisfy the condition dg(v) > dgc(v) + 1, the size
of S must be at least 6k, a contradiction to the assumption that the size of S is at most
4n+2d+ 1. Next we show that it can be assumed that SN DY = (). Suppose that S contains
an element of DY. As D" is a clique and D" N N(S) = (), if S contains one element of D"
then DY C S. This is not possible as D" has 12n + 1 elements and S has at most 4n +2d + 1
elements. Therefore, we may assume that (S U N(S)) N DY = ). This in turn implies that

S does not contain any element from Vy U R. As the offensive alliance S is non-empty, we
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have SN D? # () or SNW # (. Note that in either case, we get N(S) N (RU Vy) # 0.
Let u be an arbitrary element of N(S) N (RU Vy). If SN D® =  then clearly we have
ds(u) < dge(u) + 1 which is not possible. Therefore S N D # (). We observe that if S
contains one element of D® then it contains all elements of D®, that is, D® C S. As
D? C Sand (RUVy)NS =0, we get (RUVy) C N(S). As S is an offensive alliance, every
element u of N(S) has to satisfy the condition dg(u) > dge(u) + 1. Consider an arbitrary
vertex r; of R. If S N {w;1,wpn}t = 0 then dg(r;) = 3 and dge(r;) = 4 which is not possible
as r; does not satisfy the condition dg(r;) > dge(r;) + 1. This implies that, for each i € [n],
S contains at least one element from {ws,wp}. Since |S| < 4n +2d + 1 and D> C S, S
contains exactly one element from {w;1, w2} for each i. Define a string y = y[1]y[2] ... y[n],
where y[i] = 1 if wy € S and y[i] = 2 if wp € S. We claim y is a central string. Assume,
for the sake of contradiction, that there exists a string x; such that dgy(z;,y) > d. In this
case, we see that dg(v,,) < 3n+2d+1)+n—d < 4n+d and dge(v,;) > 4n+ d. Therefore,
ds(vy,) < dge(vy;) + 1, which is a contradiction. O

7.5 Faster FPT algorithms parameterized by vertex cover

number

We know that both OFFENSIVE ALLIANCE and STRONG OFFENSIVE ALLIANCE admit FPT
algorithms [92] when parameterized by the vertex cover number of the input graph. The
algorithms in [92] use INTEGER LINEAR PROGRAMMING, and thus their dependency on
the parameter may be gigantic. The reason is this. The OFFENSIVE ALLIANCE problem is
mapped to an ILP with at most 27%(®) many variables where vc (G) is the vertex cover number
of the input graph. It is proved in [47] that p-VARIABLE INTEGER LINEAR PROGRAMMING
OPTIMIZATION (p-OPT-ILP) can be solved using O(p*°*+°®) . L . log(M N)) arithme(zctjc
gve
)

time algorithm.

operations and space polynomial in L. Thus the algorithm in [92] requires O*((2V°(G))O(
time. The natural question would be whether they admit O*(ve(G)°V<®)
We answer this question with the following theorem.

Theorem 7.5.1. OFFENSIVE ALLIANCE can be solved in time O*(vc(G)°7®) where vc(G)

is the vertex cover number of the input graph G.

Proof. Let C' be a vertex cover of GG of size vc(G). Note that C' forms an offensive alliance.
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This is because N(C') is an independent set and every vertex of N(C) has no neighbours
in C° and has at least one neighbour in C. Therefore we have de(v) > dee(v) + 1 for all
v € N(C), and hence C' is an offensive alliance. This implies that the size of minimum
offensive alliance is at most vc(G). In [50], it was proved using branching technique that
OFFENSIVE ALLIANCE problem parameterized by solution size admits a O(n2k(2k)F=1).
This implies that, we have an algorithm with running time O*(vc(G))ve(©®. O

The arguments in the proof of Theorem is also applicable to STRONG OFFENSIVE
ALLIANCE as long as the input graph G has minimum degree at least two. As a direct
consequence of Theorem we have the following corollary.

Corollary 7.5.2. STRONG OFFENSIVE ALLIANCE can be solved in time O%(ve(G)°@)

where vc(G) is the vertex cover number of the input graph G with §(G) > 2.

7.6 FPT algorithm parameterized by vertex integrity

In this section, we present an FPT algorithm for OFFENSIVE ALLIANCE parameterized by

vertex integrity. Lets begin by recalling the definition of vertex integrity.

Definition 7.6.1. The vertex integrity of a graph G, denoted vi(G), is the minimum integer
k satisfying that there is X C V(G) such that | X| + |V(C)| < k for each component C' of
G — X. We call such X a vi(k)-set of G.

Theorem 7.6.1. OFFENSIVE ALLIANCE is fized-parameter tractable when parameterized by

the vertex integrity.

Proof. Let G = (V, E) be a graph and X C V be a vi(k)-set of G. Then C = G — X is a
collection of disjoint components, that is C = {Cy, Cs, ...} such that | X| +|C;| < k for all 7.
We know C can be partitioned into equivalent classes Cy,Cs,.... Let C; be a representative
of the equivalence class C; and let v € ;. Note that v has neighbours only in X U Cj, that
is, N(v) € X UCj. We next guess P = SN X and @ = X N N(S), where S is a smallest
offensive alliance. There are at most 3/%I < 3* candidates for (P, Q) as each member of X
has three options: either in P, @ or X \ (P UQ). We reduce the problem of finding the rest
of S to an integer linear programming (ILP). We say A; C C} is a valid selection from Cj if
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and only if each u € N(P U A;) N () satisfies

d(u) +1
2

N(A) N (X N\ (PUQ)) =

[Npua,(u)| 2

Every component in C; contributes some vertices in the solution S. We introduce a variable
y(A;) which denotes the number of components in the equivalence class C; that contribute A4,

(up to isomorphism) to S. In the following, we present ILP formulation of offensive alliance.

IPI+> ) y(A)|A).

1 ACC

The objective is to minimize

For each equivalence class C;, we have the following constraint Y. y(A;) = |C;| where |C|
A CC
denotes the number of components in C;. Next we add constraints to make sure that each

vertex u € @) satisfies |[Ng(u)| > |Nge(u)| + 1, that is, Ng(u) > %. Note that

[Ns(u)| = [Np(u)| +) Y [Na(u)] x y(4).

1 ACC
Therefore for each vertex u € (), we have the constraint:

N+ 3 3 W | x (A > WL

A CCy

In the following, we present ILP formulation of offensive alliance problem, where P and @)

are given:
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Minimize [P|+) Y y(4)| Al
I ACC
Subject to
d oy =lal Vi
A CCy
NA)N X\ (PUQ) =0 VI VACG
d(u)+1
‘NPUAl(u)‘ZT Vl,VAlgOl,VUEN(PUAl)ﬂOZ,
d(u)+1
|Np(u |+XI:A2C; INa()] x y(A) > =——  VueQ

In the formulation for OFFENSIVE ALLIANCE, we have at most 20(:) variables. The
value of objective function is bounded by n and the value of any variable y(A;) in the integer
linear programming is bounded by n. Each constraint can be represented using at most
029 (k%) )logn) bits. Lemma [2.3.2 implies that we can solve the problem with the guess
(P,Q) in FPT time. There are at most 3* choices for (P, Q) and the ILP formula for a given
guess can be solved in FPT time. Thus Theorem holds.

7.7 Classical lower bounds under ETH

The brute-force approach to find a minimum size offensive alliance is to evaluate all 2" — 1
possible non-empty subsets and check for each one whether it is an offensive alliance or not.
Checking if a specific set of vertices is an offensive alliance takes O(n?) time. So the running
time of this algorithm is O*(2"). We use O* notation to hide polynomial factors (in the
input size) in the running time. In this section, we give an algorithmic lower bound for
the OFFENSIVE ALLIANCE problem using exponential time hypothesis. In computational
complexity theory, the exponential time hypothesis (ETH) is an unproven computational
hardness assumption that was formulated by Impagliazzo and Paturi (1999). The hypoth-
esis states that 3-SAT cannot be solved in subexponential time in the worst case. While
OFFENSIVE ALLIANCE can be solved in time O*(2") for general graphs on n vertices, we

now prove that the existence of algorithms with running time 2°™ is unlikely. In order
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to prove that a too fast algorithm for OFFENSIVE ALLIANCE contradicts ETH, we give a
reduction from VERTEX COVER in graphs of maximum degree 3 and argue that a too fast
algorithm for OFFENSIVE ALLIANCE would solve VERTEX COVER in graphs of maximum
degree 3 in time 2°™. Johnson and Szegedy [85] proved that, assuming ETH, there is no
algorithm with running time 2°™ to compute a minimum vertex cover in graphs of maximum

degree 3.

7.7.1 OFFENSIVE ALLIANCE on bipartite graphs

Theorem 7.7.1. Unless ETH fails, OFFENSIVE ALLIANCE does not admit a 2°(®) algorithm,

even when restricted to bipartite graphs.

Proof. We give a linear reduction from VERTEX COVER in graphs of maximum degree 3
to OFFENSIVE ALLIANCE, that is, a polynomial-time algorithm that takes an instance of
VERTEX COVER and outputs an equivalent instance of OFFENSIVE ALLIANCE whose size
is bounded by O(n). Let (G, k) be an instance of VERTEX COVER, where G = (V, E) has
maximum degree 3. We construct an equivalent instance (G’, k') of OFFENSIVE ALLIANCE
the following way. See Figure for an illustration. Take two distinct copies Vy, Vi of

i W Ey

U1

vy €2 w3

G

Figure 7.7: An illustration of the reduction from VERTEX COVER to OFFENSIVE ALLIANCE
in Theorem [7.7.1

V = {v1,v9,...,0,}, and let v* be the copy of v € V in V;. We introduce the vertex set Ej
into G', where Fy = {e1,..., e}, the edge set of G. We make v) adjacent to e; if and only
if v; is an endpoint of e; in G. We make v{ adjacent to v} for all 7. Next, introduce five new
vertices a,b,c,d,e. For each x € {a,b,c,d, e}, introduce a set V,, of 4k’ vertices and make z

adjacent to every vertex of V.. Moreover, we make vertex a and e adjacent to every vertex of
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Ey and make b and ¢ adjacent to every vertex of V;. We also make d adjacent to every vertex

of {a,b,c,e}. Note that G’ is a bipartite graph with bipartition {d}UV; UE, |J V. and
z€{a,b,c}

{a,b,c,e} UV UVy. We set k' =k + 5. Clearly, the size of G’ is bounded by O(n).

We claim that (G, k) is a yes-instance of VERTEX COVER if and only if (G', k') is a yes-
instance of OFFENSIVE ALLIANCE. Suppose (G has a vertex cover S of size at most k. We
show that D = {v° € Vj | v € S} U{a,b,¢c,d, e} is an offensive alliance of size at most &’ in

G'. Wesee N(D)=E,uV; |J V. Itisclear that for eachv e Vy, |J V., we have
z€{a,b,c,d} z€{a,b,c,d}
dp(v) > dpe(v)+1. Each v € Ej has at least three neighbours in D, more precisely, a, e and

at least one neighbour in V4. This implies that for each v € Ey, we have dp(v) > dpe(v) + 1.

Conversely, assume that GG’ admits an offensive alliance D of size at most k' = k + 5.
Note that the vertices a, b, c,d and e cannot be part of the set N(D) as each of them has
degree 4k’; otherwise the size of D will exceed k’. We now show that {a,b,c,d,e} C D. By
definition, offensive alliance cannot be empty; therefore it must contain a vertex from V(G’).

Case 1: Suppose D contains an arbitrary vertex of U V... Without loss of generality,
ze{a,b,c,d,e}
assume that D contains an arbitrary vertex of V,. Since a ¢ N(D), it implies that a € D.

Since a € D, we get b, ¢, d and e also lie in D as otherwise {b, ¢, d,e} C N(D). Therefore, if
any vertex from the set {a,b,c,d, e} is in D, it implies that the whole set is in D.

Case 2: Suppose D contains an arbitrary vertex of V4. It implies that b, ¢ are in D and
therefore {a,b,c,d,e} C D. Suppose D contains an arbitrary vertex of Ey. It implies that
{a,e} C D and therefore {a,b,c,d,e} C D. Suppose D contains v° from V. This implies that
vt € V1 isin N(D). If both b and ¢ are outside D then we have dp(v') < dpe(v') + 1, which
is a contradiction. This implies that either b or ¢ is in D and therefore {a, b, c,d,e} C D.

Now since {a, b, c,d,e} C D, a vertex e in Fy will be either in N(D) or D. If e € D, then
we pick an arbitrary neighbour of e in V) and put it in D and remove e from D. Therefore,
staring with an arbitrary offensive alliance D, we can transform it into another offensive
alliance such that D N Ey = ), that is, Ey € N(D). As each e € Ej has to satisfy the
condition dg(e) > dge(e) + 1, we must have a set S C V; of size at most &k in D such that
every vertex in Fjy has at least one neighbour in S. This implies that S is a vertex cover of

size at most k£ in G. OJ
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7.7.2 STRONG OFFENSIVE ALLIANCE on apex graphs

Recall that a planar graph is a graph that can be embedded in the plane, that is, it can be
drawn on the plane in such a way that its edges intersect only at their endpoints. In other
words, it can be drawn in such a way that no edges cross each other. An apex graph is a
graph that can be made planar by the removal of a single vertex. The deleted vertex is called

an apex of the graph.

Theorem 7.7.2. STRONG OFFENSIVE ALLIANCE admits a O*(2°0/r1e™)) qlgorithm on
apex graphs.

Proof. Note that the treewidth of any apex graph with n vertices is bounded by O(y/n). In
[70], a polynomial time algorithm is given to solve offensive alliance problem on bounded
treewidth graphs with running time O*(2*n°“)) where w denotes the treewidth of the input
graph. This algorithm can be used to obtain an algorithm with running time O*(20(vV71logn))
for apex graphs. n

While STRONG OFFENSIVE ALLIANCE can be solved in time O*(2°(V?1°8™) for apex graphs
on n vertices, we now prove that the existence of algorithms with running time 2°V™ is

unlikely.

Theorem 7.7.3. Unless ETH fails, the STRONG OFFENSIVE ALLIANCE problem does not

admit a 2°0V™ algorithm even when restricted to apex graphs.

Proof. We give a linear reduction from PLANAR DOMINATING SET to STRONG OFFEN-
SIVE ALLIANCE, that is, a polynomial-time algorithm that takes an instance of PLANAR
DOMINATING SET on n vertices and m = O(n) edges, and outputs an equivalent instance of
STRONG OFFENSIVE ALLIANCE whose size is bounded by O(n). Let (G, k) be an instance of
PLANAR DOMINATING SET. Without loss of generality, we assume that GG is connected. We
construct an equivalent instance (G', k') of STRONG OFFENSIVE ALLIANCE in the following
way. See Figure [7.9 for an illustration.

To construct graph G’, we start with graph G. For every edge e € F(G), we add one
parallel edge € with the same endpoints. We subdivide each edge e € E(G) and the sub-

division vertex for e is denoted by v.. For each e € E(G), we introduce a new vertex h,
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Figure 7.8: The reduction from PLANAR DOMINATING SET to STRONG OFFENSIVE AL-
LIANCE in Theorem [7.7.3]

and make it adjacent to v.. We introduce two new vertices x and x’. Finally, we add a
set VU of 4(m + k + 2) vertices and make z and 2’ adjacent to every vertex of V. Lastly,

we make = adjacent to every vertex of V(G)U |J {ve,he}. This completes the construc-
e€E(Q)
tion of G'. Set k' = m~+k+2 We observe that G'—x is planar. Therefore, G’ is an apex graph.

Formally, we claim that G has a dominating set of size at most k if and only if G’ has a
strong offensive alliance of size at most k’. Suppose G admits a dominating set D of size at

most k. We claim that S = DU {z,2’} U |J {v.} is a strong offensive alliance of size at

e€E(Q)
most k'. It is easy to see that |S| < k’. We note that N(S) = | {h.}uU(V(G)\ D)UVLE.
e€E(GQ)
Let v be an arbitrary element of N(S). If v is an element of |J {h.} or V7, then it has
e€E(GQ)

two neighbours in S and no neighbours in S¢, so we have Ng(v) > Ngc(v) 4+ 2. Suppose v is
an element of V(G)\ D. If v has degree d in G then d¢/ (v) = 2d+ 1. Since D is a dominating
set, v € V(G) \ D has at least one neighbour in D. Thus v has at least d + 2 neighbours in
S and at most d — 1 neighbours outside S. This implies that Ng(v) > Nge(v) + 2. Therefore
S is a strong offensive alliance.

In the reverse direction, suppose now that S is a strong offensive alliance of size at most k’.
We first show that {z,2'} C S. As both z and 2’ have degree more than 2k’ we claim that
x and 2’ cannot be in N(S). Suppose, for the sake of contradiction, that x € N(S). Then
x must satisfy the condition Ng(x) > Nge(x) + 2. This implies that the size of S is greater

than k', a contradiction to the assumption that S is a strong offensive alliance of size at
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most &’. As S is a non-empty set it contains at least one vertex of G’. Case 1: S contains z;
then we are done. Case 2: S contains an element of V(G') \ {z}. As z is adjacent to every
other vertex of G', z € N(S). Since we know x cannot be in N(S), so we must have x in S.
Once z is in 9, the vertices of V7' are in N(S). The vertices v of V' satisfy the condition
ds(v) > dge(v) + 2, only if 2’ € S.

Next we claim that for each e € F(G) if v, is not in S then h, must be in S. Suppose, for
the sake of contradiction, that h. is not in S. Then h, € N(S) as h. is adjacent to x and
x € S. Note that h. has one neighbour = in S and one neighbour v, in S¢. Therefore we
get Ng(he) < Nge(h) + 2, a contradiction to the assumption that S is a strong offensive
alliance. This proves the claim.

Therefore for each e € E(G), either v, € S, he € S or both v, h, are in S. Starting from
an arbitrary strong offensive alliance S, we can always construct another strong offensive

alliance S’ such that |J {v.} C€ 5" and |S’| < |S|. We construct S’ from S as follows. For
ecE(G)
each e € E(G) do the following: if S contains both v, and h,, remove h; if S contains only h,,

replace it by ve; if S contains v, do not make any changes. Clearly |S’| < |S|. We claim that

S’ is also a strong offensive alliance. Note that N(S5") = (N(S)\ U {ve}> U U {he} Tt
e€E(G) ecE(G)
is easy to see that, for each e € E(G), we have Ng(h.) > Ngc(h.) + 2. For any other vertex

v € N(S5), we have Ng/(v) > Ng(v). This proves the claim that S’ is a strong defensive

alliance. Therefore we obtain a strong offensive alliance S’ of size at most k' such that

{z,2’}U U v.C 5"
e€E(Q)
Now we claim that S’ N V(@) is a dominating set of size at most k in G. Note that |S’ N

V(GQ)| < k as S’ contains {z,2'}U |J v and |S’| < k+m+ 2. Let us consider any vertex
e€E(Q)
u € V(G). If dg(u) = d then der(u) = 2d + 1. If u € "N V(G) then it is dominated

by itself. Therefore, let us assume that v € V(G) \ S’. Suppose u has no neighbours in
S"NV(G). Then it would imply that de(u) = d+ 1 and dg<(u) = d. In this case, we see
that dg/(u) < dgie(u) + 2 which is a contradiction. This implies that u has a neighbour in
S"NV(G). That is, every vertex of G is dominated by the set S" NV (G). O

7.8 NP-completeness results

In this section, we prove that the OFFENSIVE ALLIANCE problem is NP-complete, even

when restricted to split, chordal and circle graphs.
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7.8.1 Split and Chordal Graphs

A graph G is called chordal if it does not contain any chordless cycle of length at least four.
Split graphs are a subclass of chordal graphs, where the vertex set can be partitioned into

an independent set and a clique. We now prove the following theorem.

Theorem 7.8.1. The OFFENSIVE ALLIANCE problem is NP-complete, even when restricted
to split or chordal graphs.

Proof. 1t is easy to see that the problem is in NP. To show that the problem is NP-hard
we give a polynomial reduction from VERTEX COVER in graphs of maximum degree 3. Let
(G, k) be an instance of VERTEX COVER, where G has maximum degree 3. We construct
an equivalent instance (G, k") of OFFENSIVE ALLIANCE the following way. See Figure

for an illustration. The vertex set of G’ is defined as follows:

‘/node ‘/edge
U1
61/;\62
V2 e, Us X
3
Y
G

G/

Figure 7.9: An illustration of the reduction from VERTEX COVER to OFFENSIVE ALLIANCE
in Theorem [7.8.1

1. For every v € V(G), we introduce v into G'. Set Vioae = V(G).
2. For each e € E(G), we introduce a vertex e into G'. Let Vigge = {€; | €; € E(G)}.
3. We introduce a set Y = {y1,y2, ..., Yms1} of m + 1 new vertices into G'.

4. Moreover, introduce a set X = {x1,..., Zapm+m)} of 4(n + m) new vertices into G’
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We now create the edge set of G'.

1. For every v; € Viode and e; € Vigge, make v; adjacent to e; if and only if v; is an

endpoint of e; in G.
2. Make the the set Vigee UY a clique in G'.

3. Finally, we make every vertex of X adjacent to every vertex of Y.

Note that Vjoqe UX forms an independent set where as the vertices in Vg, UY form a clique.
Therefore, G is a split graph. We set &' = k +m + 1.

Formally, we claim that G has a vertex cover of size at most k if and only if G’ has an
offensive alliance of size at most k’. Assume first that G admits a vertex cover S of size at
most k. Consider D = SUY. Clearly, |D| < k. We claim that D is an offensive alliance
in G’. Note that N(D) = Viqge U X. For each x € X, we have dp(z) > dpe(x) + 1 as all
its neighbours are inside D. Each e € Vg4 has at least m + 2 neighbours in D and at most
m + 1 neighbours, including itself, outside D. This implies that D is an offensive alliance of

size at most k' in G".

For the reverse direction, let D be an offensive alliance of size at most &' in G'. We first
show that Y C D. It is easy to note that Y N N(D) = () as otherwise each v € YN N (D) has
to satisfy the condition dp(v) > dpe(v) 4+ 1 which requires more than £’ vertices in D. Since

D is a non-empty offensive alliance, it must contain a vertex from the set V04 UVeqge UX UY'.

Case 1: Suppose D contains a vertex v from Veqee UX UY. Then v has at least one neighbour
inY. As Y N N(D) = 0, therefore we get Y C D.

Case 2: Suppose D contains a vertex v from Vioge. Let e € Vigee be a neighbour of v
in G'. Then e could be either in D or in N(D). If e is in D, then Case 1 implies that
Y C D. Suppose e is in N(D). Then e has to satisfy the condition dp(e) > dpc(e) + 1.
Note that the neighbours of e in G’ are the vertices of Y U Vegqe \ {€} and two endpoints
of e in Viode, thus dgr(e) = 2m + 2. In order to satisfy the condition dp(e) > dpe(e) + 1,

vertex e requires at least one vertex from Y to be inside D. Again Case 1 implies that Y C D.
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Since the size of D is at most m + k+ 1 and Y C D, it can contain at most k vertices
besides the vertices in Y. Given an offensive alliance D, we can construct another offensive
alliance D’ such that |D'| < |D| and D' N (Vegge U X) = 0, in the following way. For each
e € Veage N D, we replace e by an arbitrary neighbour of e in Vjoqe. If a neighbour of e is
already present in D then just remove e and do not add any new vertex. We also remove all
the vertices of X from D. The modified D is our D’. Next we argue that D’ is an offensive
alliance. Since Y C D’ and D' N (V. U X) = 0, we have N(D') = Vigge U X. Let v be an
arbitrary element of N(D').

Case 1: If v € X then all its neighbours in G’ are in D’. So v trivially satisfies the condition
dp(v) 2 dpre(v) + 1.

Case 2: Suppose v € N(D) N Vegge. We know Vegge € D U N(D). We observe that for the
vertices in N (D) N Veqge, we only increase their number of neighbours in D’. Therefore, every
vertex v of N (D) N Veqge satisfy the condition dp/(v) > dpre(v) + 1.

Case 3: Suppose v € D N Veqge. Every vertex v of Vegge N D has at least one neighbour from
Viode inside D’ by the construction of D’. Clearly, each vertex of Veqee N D has at least m + 2
neighbours inside D’ and at most m + 1 (including itself) outside D'.

This shows that D’ is an offensive alliance. Note that Vegee € N(D’). Therefore, every
e € Veage satisfies the condition dp/(e) > dpre(e) + 1, which requires at least one neighbour
of e from V,qe to be inside D’. Therefore D' NV, 4. forms a vertex cover of size at most k

in G. This proves that (G, k) is a yes instance. O

7.8.2 Circle graphs

A circle graph is an undirected graph whose vertices can be associated with chords of a
circle such that two vertices are adjacent if and only if the corresponding chords cross each
other. Here, we prove that the OFFENSIVE ALLIANCE problem is NP-complete even when
restricted to circle graphs, via a reduction from DOMINATING SET. It is known that the
DOMINATING SET problem on circle graphs is NP-hard [8§].

Theorem 7.8.2. The OFFENSIVE ALLIANCE problem on circle graphs is NP-complete.

Proof. 1t is easy to see that the problem is in NP. To show that the problem is NP-hard
we give a polynomial reduction from DOMINATING SET on circle graphs. Let (G, k) be an
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c2 G G}
b

(a) (b)

Figure 7.10: (a) Graph G and its circle representation. (b) The graph G’ produced by the
reduction algorithm. Note that every orange vertex is adjacent to a set of 2r vertices, which
are not shown here.

instance of DOMINATING SET, where G is a circle graph. Suppose we are also given the circle
representation C' of G. Without loss of generality, we assume that there are no one degree
vertices in G. We construct an instance of (G, r) of OFFENSIVE ALLIANCE as follows (see
Figure. Set 7 = 2m + k, where m is the number of edges in G. For every v € V(G), we
introduce two cliques C! and C? where C! has L@j nodes and C? has f‘ig—”)} nodes; make
v adjacent to every vertex of C} U C}; for every x € Cy U CZ, introduce a set V-, of 2r new
vertices and make z adjacent to every vertex of V. The vertices of V), are not shown in
Figure [7.10p. We start at an arbitrary vertex of the circle representation C' of G and then
traverse the circle in a clockwise direction. We record the sequence in which the chords are
visited. For example, in Figure , if we start at the red vertex on the circle, then the
sequence in which the chords are visited, is a, b, d, ¢, a, d, b, c. Note that every vertex appears
twice in the sequence as every chord is visited twice while traversing the circle. Thus we get
a sequence S of length 2n where n is the number of chords in C'. We use the sequence to
connect 2n newly added cliques. For every consecutive pair (u,v) in the sequence S, put an
edge between a vertex of C! (resp. C?) and a vertex of C! (resp. C?) if both u,v appear
for the first time (resp. second time) in the sequence; put an edge between a vertex of C!
and a vertex of C? if u appears for the first time and v appears for the second time in the
sequence. These edges are shown in orange in Figure|7.10b. This completes the construction

of graph G'. Now we show that G’ is indeed a circle graph.
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(a) (b)

Figure 7.11: (a) The circle representation for the first operation. Let d(v) = 6. For v, we
introduce C! in G’, and make v adjacent with every vertex of C!. The circle representation
of v, C! and their adjacency are shown here. (b) The circle representation for the second
operation with r = 1.

Figure 7.12: A circle representation of the graph G’ in Figure We do not shown the
parallel chords correspond to 2r vertices adjacent to every vertex in each clique.

In the reduction algorithm, we have three operations: (i) For every v € V(G), we introduce
two cliques C! and C? and make v adjacent to every vertex of C! and C2. This operation
can be incorporated in the circle representation by introducing L@J intersecting chords at
one end of the chord corresponds to v and [@1 intersecting chords at the other end of
the chord corresponds to v. See Figure for an illustration. (ii) For every vertex x in
clique, we introduce a set of 2r new vertices and make x adjacent to each of them. This
operation can be easily incorporated in the circle representation by introducing 2r parallel
chords intersecting the chord corresponds to x. See Figure|7.11p. (iii) For every consecutive
pair (u,v) in the sequence S, we put an edge between a vertex of C! and a vertex of C}. This
is incorporated in the circle representation by making the last chord (in clockwise direction)

of C! intersect with the first chord (in clockwise direction) of C'!. This is demonstrated in

Figure

Formally, we claim that G has a dominating set of size at most k if and only if G’ has an

offensive alliance of size at most r. Assume first that G admits a dominating set S of size
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at most k. Consider
D= |J vchuv(chus.
veV(G)

Clearly |D| < r. We claim that D is an offensive alliance in G'. Clearly

ND)=v@&)\S | U v

veV(Q) zeCluC?

Each v € U VI satisfies dp(u) > dpe(u) + 1. For u € V(G)\ S, if dg(u) = d
VeV (G) zeCiuc?
then in G’ we have dp(u) > d+ 1 and dpe(u) < d—1. Thus D is an offensive alliance of size

at most r in G'.

Conversely, suppose G’ admits an offensive alliance D of size at most r. First, we claim
that
U viehuves) co.
veV(Q)
It is to be noted that any offensive alliance D of size at most r cannot contain a vertex of

degree more than 2r in its neighbourhood N (D). As every vertex of |J V(C})UV(C?)
veV(G)
has degree more than 2r, we have that N(D)n |J V(CH)UV(C?) = 0. Since D is a
veV(QG)
non-empty offensive alliance, it must contain a vertex from G’. Suppose D contains a vertex

z from V(C!). As z is adjacent to all other vertices in C!!, the remaining elements of V(C!)
are in N(D). We know N(D) cannot contain any element of V(C!), therefore we have
V(CY) € D. Similarly, as N(D) cannot contain any element of |J V(C!)U V(C?), it

veV(Q)
implies that |J V/(C!)UV(C?) C D. This proves the claim.
veV(Q)
Note that the total number of vertices in  |J V(CH)UV(C?) is 3. d(v) = 2m and the
veV(Q) veV
size of D is at most 2m + k. Therefore, besides the vertices in | J V(C})uV(C?), D can
veV(Q)

include at most k vertices from V(G). The remaining vertices of V(G) are in N(D). Each
v € V(G)NN(D) needs exactly one neighbour from V(G) N D, in addition to its neighbours
in C! U C?, in order to satisfy the condition dp(v) > dpe(v) + 1. Therefore, D NV (G) is a

dominating set of size at most k£ in G. n
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7.9 Closing Remarks and Future Directions

In this work we proved that the OFFENSIVE ALLIANCE problem is NP-complete even when
restricted to bipartite, chordal, split and circle graphs. We proved that the OFFENSIVE
ALLIANCE problem is W|1]-hard parameterized by a wide range of fairly restrictive structural
parameters such as the feedback vertex set number, treewidth, pathwidth, and treedepth
of the input graph thus not FPT (unless FPT = W][1|). We thereby resolved an open
question stated by Bernhard Bliem and Stefan Woltran (2018) concerning the complexity of
OFFENSIVE ALLIANCE parameterized by treewidth. This is especially interesting because
most “subset problems” that are FPT when parameterized by solution size turned out to be
FPT for the parameter treewidth [33], and moreover OFFENSIVE ALLIANCE is easy on trees.

(6)°0<@)) where ve(G)

On the positive side we proved that it can be solved in time O*(vc
is the vertex cover number of the input graph, and the problem admits an FPT algorithm
when parameterized by vertex integrity of input graph. We gave lower bound based on ETH
for the time needed to solve the OFFENSIVE ALLIANCE problem; we proved that it cannot
be solved in time 2°0™ even when restricted to bipartite graphs, unless ETH fails. We list

some natural questions that arise from the results of this study:

e W|t|-membership of DEFENSIVE ALLIANCE problem on graphs of bounded treewidth.

e Does OFFENSIVE ALLIANCE parameterized by vertex cover number admit a single

exponential algorithm or can one show a lower bound with matching time complexity?

e Does OFFENSIVE ALLIANCE admit polynomial-time algorithms on some special classes
of intersection graph family such as interval graphs, circular arc graphs, unit disk

graphs, etc?

e Determine parameterized complexity of OFFENSIVE ALLIANCE problem when param-
eterized by other structural parameters such as twin cover, cluster vertex deletion

number and modular-width.
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Chapter 8

F-Free Edge Deletion

8.1 Introduction

Given a graph G = (V, E) and a set F of forbidden subgraphs, we study the /-FREE EDGE
DELETION problem, where the goal is to remove a minimum number of edges such that the
resulting graph does not contain any F' € F as a (not necessarily induced) subgraph. Enright
and Meeks (Algorithmica, 2018) gave an algorithm to solve F-FREE EDGE DELETION whose
running time on an n-vertex graph G of treewidth tw(G) is bounded by 2°00FW(E) )y if every
graph in F has at most r vertices. We complement this result by showing that /-FREE EDGE
DELETION is W|[1]-hard when parameterized by tw(G) + |F|. We also show that F-FREE
EDGE DELETION is W|2|-hard when parameterized by the combined parameters solution
size, the feedback vertex set number and pathwidth of the input graph. This chapter is
based on the paper [62].

8.2 Hardness of F-FREE EDGE DELETION parameterized
by tw(G) + |F]

Enright and Meeks [44] gave an algorithm to solve F-FREE EDGE DELETION whose run-

ning time on an n-vertex graph G of treewidth tw(G) is bounded by 2007 W(E) )y - if every
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graph in F has at most r vertices. In this section, we complement this result by showing
that F-FREE EDGE DELETION is W[1]-hard when parameterized by tw(G) + |F|. To show
W|[1]-hardness of F-FREE EDGE DELETION, we reduce from the following problem, which
is known to be W|[1]-hard parameterized by the treewidth of the graph [123]:

MINIMUM MAXIMUM OUTDEGREE

Input: An undirected graph G = (V, E), an edge weighting w : F(G) — Z* given in
unary and a positive integer r.

Question: Is there an orientation of the edges of G such that, for each v € V(G), the

sum of the weights of outgoing edges from v is at most r?

The weighted degree d,,(u; G) of a vertex w € V' is defined as >  w(u,v). The weighted
vENG(u)

mazximum degree A, (G) of G is defined as max dy(u; G). In this section, we prove the
ue

following theorem:

Theorem 8.2.1. The F-FREE EDGE DELETION problem is W/[1]-hard when parameterized
by tw(G) + | F|.

Proof. Let I = (G = (V, E,w),r) be an instance of the MINIMUM MAXIMUM OUTDEGREE
problem.

Construction: We construct an instance I’ = (G', k, F) of F-FREE EDGE DELETION the
following way (see Figure .

1. For each edge (u,v) € E(G), we introduce the following sets of new vertices V,,, =

{ul, ...  Uny ) o Vaw = {045 -+ ,ug(uyv)}, Vou = {0¥, . .. ,vﬁ(w)} and V), = {v}*, ... ,vﬁj(u’v)}.

We make u (resp. v) adjacent to all the vertices in V,, U V) (resp. V,, UV/,). Let
By = {(u,x) | x € Vuv}, By = {(u,x) | x € Vév}, By = {(v,x) | x € Vw} and

Bl ={(v,2) |2 € V,,}.

2. For every vertex u € V(G), we also add a set V= of A, (G) — dy,(u; G) many vertices

and make them adjacent to u.

3. Arrange the vertices of G in a linear order. We define two sets of pairs of vertices:

Cr = {1, o)} | (w,0) € B(G),1 < i < wlu,v)}

U {(u;vﬁvzl‘q—f—l)ﬂ (ug(u,vwvllu) ’ (U,U) < E(G)> 1<i< ’LU(U,’U) o 1}7
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Ca = {1, o)} | (u,v) € B(G),1 < < wlu,v)}

U {2 050), (v | (w0) € B(G),1 < i S wluyv) — 1},

In the definition of C; and Cj, for each (u,v) € E(G), consider u appears before v in

the linear ordering.

4. Let w= > w(e)and N =n+ 3w + 1. For every pair of vertices (v, v™) € C}, we
e€E(G)
add a “blue" path P, ,m of length 4N — 2 joining «'¥ and v™, whose internal vertices

are new. Similarly, for every pair of vertices (u”,v") € Cy, we add a “red" path Pyv ,u

of length N joining " and v*, whose internal vertices are new.

5. We set k = w and F = {Sa,(G)+r+1, Csn42} where Csyo is the cycle graph of order
5N + 2 and Sa,(G)4r+1 is the star graph of order A, (G) +r + 1.

Figure 8.1: The reduction from MINIMUM MAXIMUM OUTDEGREE to F-FREE EDGE DELE-
TION in Theorem (a) An undirected graph G with edge weights and r = 3. (b) The
graph G’ produced by the reduction algorithm. The red dashed lines represent red paths of
length N, and the blue dashed lines represent blue paths of length 4N — 2.

Clearly I’ can be computed in polynomial time. In the I’ instance, we have |F| = 2. We now
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show that the treewidth of G’ depends only on the treewidth of G. We do so by modifying

an optimal tree decomposition 7 of GG as follows:

e For every edge (u,v) of G, there is a node t in 7 whose bag B contains u and v. We
observe that the gadget replacing the edge (u,v) € E(G) is the union of two vertex-
disjoint cycles after removing the vertices u and v. As any cycle has treewidth two,
we can construct a tree decomposition 7, of this gadget of width four. Now, we make

the node t adjacent to an arbitrary node of 7,,.

e For cach u € V(G), we take an arbitrary node in 7 whose bag B contains u; add to
this node a chain of nodes Ny, Ny, ..., Njyo| such that the bag of N; is BU {z;} where
x; € VuD.

This implies that the treewidth of G’ is at most treewidth of G plus four.

Correctness: Now we show that our reduction is correct. That is, we prove that I =
(G = (V,E,w),r) is a yes instance of MINIMUM MAXIMUM OUTDEGREE if and only if
I'=(G' k,F) is a yes instance of F-FREE EDGE DELETION. Let D be the directed graph
obtained by an orientation of the edges of GG such that for each vertex the sum of the weights

of outgoing edges is at most r. We claim that the set of edges

E = U Eypu = U {(v,x) |z € VW}

(u,w)EE(D) (u,w)EE(D)

is a solution of I’. Note that (u,v) or (v,u) is a directed edge of D. Clearly, we have
|F'| = w. We need to show that G' = G’ \ E’ does not contain any forbidden graph

F € {Sa,(@)+r+1, Csn42} as a subgraph. We prove the following two claims:

Claim 8.2.1. G’ does not contain SAw(G)4r+1 aS a subgraph.

Proof. We show that every vertex in G’ has degree at most Ay(G) +r. It is clear from
the construction that if v € V(G')\V(G) then dz(v) < 3. Let wg ; and wi denote the
sum of the weights of outgoing and incoming edges of vertex v in D, respectively. Note
that dy(v;G) = wi, +w},. If v € V(G), then Ng/(v) = vl U VUV, Hence

u€Ng(v)
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der (V) = A (G) —dy(v; G)+2dy(v; G) as |[VE| = Ap(G)—dy(v;G)and | | Vi UV | =
uENg(v)
> 2w(u,v) = 2d,(v; G). If the direction of the edge between u and v is from u to v in
vENG(u)

D, then E' includes edges between v and V,,,; in other words, in G all edges between v and
Vi are deleted. Therefore,

Na@ =vou | wuvi)\ U Ve

uENG(v) (uv)eE(D

Thus the degree of v in G is given by

Az (v) = Ay (G) = dy(v; G) + 2dy(v; G) — wiy
= Ay(G) + dy(v; G) — wi,
= Ay (G) + (i, +wg ) — wiy
= Ay (G) +wiyt

This implies that dz (z) < Ay, (G)+r as w?
as a subgraph.

out <7 Therefore, G’ does not contain SAw(G)+r+1

Claim 8.2.2. G’ does not contain Csnio as a subgraph.

Proof. Targeting a contradiction, let us assume that G’ contains Csn42 as a subgraph. We
make two cases based on whether the cycle contains some original vertex u from V(G) or
not.

Case 1: Suppose the cycle includes at least one original vertex u € V(G). Let u be adjacent
to v in GG. Without loss of generality, we assume that the direction of the edge between u
and v is from u to v in D. Then the edges in E, ,u = {(v,x) | z € Vvu} are not present in
G Further, we make two subcases based on whether the cycle contains a “blue" edge or a

“red" edge. It is easy to note that G’ does not have a cycle that contains both red and blue

edges.

Subcase 1.1: Suppose the cycle starts at u € V(G) and includes at least one blue edge from a
blue path P, . Then the cycle includes all blue edges of the path P,w ,n of length 4N — 2
and reaches the vertex v*. There are many ways to return to u from v, but every path

from ™ to u in G’ includes another blue path, which makes the length of the cycle at least
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Figure 8.2: The gadget corresponds to an edge (u, v) with weight 3 in G'. The direction of the
edge between u and v is from u to v in D. Note that the edges of E, ,u = {(v, x) |z € VW}

are included in £ and hence not present in G,

8N — 2 > 5N + 2. This implies that a cycle of length 5N + 2 does not exist in this case.
Figure shows a “blue" cycle of length 8N — 2 that contains one original vertex u and
a “blue" cycle of length 8 N that contains two original vertices u and v. Clearly, there are

cycles of length larger than 8 N that contains more than two original vertices.

Subcase 1.2: Suppose the cycle starts at u € V(G) and includes at least one red edge. In this
case, the cycle begins with an edge e € E,, ,». Next, it must continue with a red edge. Observe
that if the cycle includes one red edge from P, .« then it must include all the red edges of
the path P, . of length N and reaches v". As edges in E, ,. are not present in @7, it must
take another red path of length N starting at v", and reaches a vertex in V,, ,», and finally
return to u. This way we can get cycles of length 2Ni+2 fori =1,2,..., w(u,v) — 1. Figure
Shows a cycle of length 4N + 2. Note that 2Ni+2 # 5N +1fori=1,2,..., w(u,v) — 1.
Thus we showed that a “red" cycle of length 5N + 2 does not exist.

Case 2: Suppose the cycle does not include any original vertex. Then it also does not include

any edge from |J E,pUE,wUE! . UE! .. Further, we make two subcases based
(u)EE(G) ’ ’
on whether the cycle contains a blue edge or not.

Subcase 2.1: Suppose the cycle contains a blue edge. Since the original vertices of V(G) are
not allowed, the cycle must include 2w(u, v) many blue paths of length 4N — 2 each. In this
case, for each (u,v) € E(G), one can get a “blue" cycle of length 2w(u,v)(4N —2) # 5N +2,
where w(u,v) is the weight of (u,v) in G. Figure|8.3|shows a “blue" cycle of length 6(4N —2).
Therefore, a “blue" cycle of length 5N + 2 does not exist.

Subcase 2.2: Suppose the cycle does not contain any blue edges. Since the blue edges and
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Figure 8.3: The gadget corresponds to an edge (u,v) with weight 3 in G'. Suppose the
direction of the edge between v and v is from u to v in D.

the original vertices in V(G) are not allowed, the cycle must contain only red edges. In this
case one can get a “red" cycle of length 2w(u,v)N # 5N + 2. Therefore, a “red" cycle of
length 5N + 2 does not exist. O

For the reverse direction, let £ C E(G’) be a solution for I’, that is, |[E'| = w and G'\ F’
dose not contain any F' € F = {Sa,(@)+r+1, Csn+2} as a subgraph. We now prove a crucial

property of E'.

Claim 8.2.3. For each (u,v) € E(G), the set E' contains either Ey v, Eyyu, E!, . or E!

w,u'v R

Proof. For each edge (u,v) € E(G), there are u-v paths of length 4N through the blue edges.
We call such paths the paths of type blue. Similarly, for each edge (u,v) € E(G), there are
u-v paths of length N + 2 through the red edges. We call such paths the paths of type red.
We observe that a path of type red and a path of type blue together form a cycle of length
5N + 2. See Figure a). Therefore, to avoid such a cycle, the solution must destroy either
all u-v paths of type red or all u-v paths of type blue for each (u,v) € E(G). Since the
number of edge-disjoint u-v paths of type red (resp. blue) is w(u,v), the minimum number
of edges whose deletion destroys all u-v paths of type red (resp. blue) is w(u,v). We must
add at least w(u,v) many edges to E’ for each (u,v) € E(G). As |E'| = w, it implies that
E’ includes exactly w(u,v) many edges for each (u,v) € E(G).

Case 1: Suppose that the deletion of edges in E’ destroys all u-v paths of type red and it
uses exactly w(u,v) edges to destroy all u-v paths of type red. We make two important

observations about E’:
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Figure 8.4: The gadget corresponds to an edge (u,v) with weight 3.

1. The solution £’ does not include any red edge. The reason is this. If we delete a red
edge then, there will be still at least w(u,v) many edge disjoint u-v paths of type red
left. For example, deletion of a red edge of the path Py ,u destroys the path Py ,u, but
there are still 3 edge disjoint u-v paths of type red as shown in Figure b). Thus,
deletion of three edges (including a red edge) is not enough to destroy all u-v paths of
type red. In general, deletion of w(u,v) edges (including a red edge) is not enough to
destroy all u-v paths of type red. Therefore, E' does not include red edges. It implies

that a solution must contain edges from E, v U E,, .

2. The solution E’ includes either (u,u}) or (v,v}) for all 1 <14 < w(u,v). If we remove
both (u,u}) and (v,v}") for some i, we will still have w(u,v) — 1 many edge disjoint
u-v paths of type red. For example, after removing two edges (u,u}) and (v}, v), we
still have two edge disjoint u-v paths of type red as shown in Figure ¢). Clearly,
deletion of 3 edges (including (u,u}) and (v},v)) are not enough to destroy all u-v
paths of type red. In general, deletion of w(u,v) edges, including two edges (u,uY)

and (v, v}), are not enough to destroy all u-v paths of type red.

188



Without loss of generality, we assume that (u,u!) is not part of the solution, that is, we are
not deleting (u,u}) from the graph G’. There are two paths P; = (u,uf, Py u, v}, v) and
Py = (u,uf, Py y,vy,v) that go through edge (u,uy). Figure|8.4(d) shows P and P. Since
we do not delete (u,uy) from the graph G’ and red edges cannot be deleted, we are forced
to delete both (v,v¥) and (v,vY), in order to destroy P; and P;. That is, edges (v,v}) and
(v,vy) are in E'. As (v,v¥) is in E’, by Observation 2 (u,u3) is not in E’. Again, it forces
(v,vy) and (v, vY) to be part of the solution. Applying this argument repeatedly, we see that
E’ contains E, yu. As |E, | = w(u,v), no edges from E, ,» can be part of the solution. This
shows that E’ contains either F, ,» or E, ,u.

Case 2: Suppose that the deletion of edges in E’ destroys all u-v paths of type blue. Using
the same arguments, we can prove that E’ contains either £ ., or E, ... This concludes

the proof of the claim. O

We now define a directed graph D by V(D) = V(G) and
E(D) = {(u, V) | By o B,y € E’} U {(U,u) | By or Bl C E’}.

Suppose there is a vertex z in D for which wg ; > r. In this case, we observe that x is
adjacent to more than A, (G)+r vertices in graph G’ = G'\ E'. This is a contradiction as ver-

tex x and its neighbours form the star graph Sa, (@)+r+1, Which is a forbidden graph in I'. [

8.3 Hardness of F-FREE EDGE DELETION parameterized
by k + fvs(G) + pw(G)

In this section we show that F-FREE EDGE DELETION is W|[2]-hard parameterized by
k + fvs(G) + pw(G), via a reduction from HITTING SET. In the HITTING SET problem, we
are given a universe U = {1,2,...,n}, a family A of sets over U, and a positive integer k.
The objective is to decide whether there is a subset H C U of size at most k& such that H
contains at least one element from each set in A. It is known that the HITTING SET problem

is W|2]-hard when parameterized by the solution size k [31]. We prove the following theorem:
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Theorem 8.3.1. The F-FREE EDGE DELETION problem is W|[2|-hard when parameterized
by k + fvs(G) + pw(G).

Proof. Let (U, A, k) be an instance I of the HITTING SET problem and let U = {1,2,...,n}.
We construct an instance I’ = (G, F, k') of F-FREE EDGE DELETION as follows. We first
introduce a central vertex v. For every ¢ € U, we attach to this vertex a cycle C; of length
2i + 2. See Figure Note that C4,Cs,...,C, have only one vertex v in common. We

Figure 8.5: The graph G of the F-FREE EDGE DELETION problem instance constructed
in the reduction of Theorem [8.3.1] for n = 3.

define GG as follows
V(G) = JV(C) and E(G)=|]E(G).
iU €U

We observe that the graph G contains a unique cycle C; of length 2i 4+ 2 for each i € U.
Clearly, {v} is a feedback vertex set of G. The feedback vertex set number of G is 1. The
pathwidth of G is 2. As G — v is disjoint union of paths, G — v has a path decomposition P
of width 1. We add v in each bag of P to get a path decomposition of G of width 2. Thus
its pathwidth is less than or equal to 2. On the other hand, G contains cycle, thus pathwith
of G is greater than or equal to 2. Therefore the pathwidth of G is 2. Now, we define a
family F of forbidden subgraphs. For every set A € A, we add a graph F4 in F, where Fy

is defined as follows:
V(Fa) = |JV(Ci) and E(Fa) =] E(C)).
i€A icA

We take k' = k. Next, we show that [ is a yes instance if and only if I’ is a yes instance. Let
H be a solution for the instance I. We see that by deleting one arbitrary edge from every

cycle C;, i € H, we can avoid all the forbidden graphs in F. Therefore, we have a solution
E’' C E(G) for the instance I’ such that |E'| < k.
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Conversely, suppose E' C E(G) with |E’| < k is a solution for the instance I'. We see
that H = {i | E(C;) N E" # 0} is a hitting set for the instance I. Furthermore, |H| < k as
E'| < k. O

Observe that the constructed instance in the previous theorem is an outerplanar bipartite

graph. Therefore we get the following result:

Corollary 8.3.2. F-FREE EDGE DELETION is W|2]-hard when parameterized by k +
fvs(G) + pw(G), even when restricted to outerplanar bipartite graphs.

8.4 Closing Remarks

The main contributions in this chapter are that F-FREE EDGE DELETION is W]1]-hard
when parameterized by |F| + tw(G) and F-FREE EDGE DELETION is W[2]-hard when
parameterized by k + fvs(G) + pw(G).
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Chapter 9

The 7}, -free edge deletion problem

9.1 Introduction

Given an undirected graph G = (V| E) and two integers k and h, we study 7, +1-FREE EDGE
DELETION, where the goal is to remove at most k& edges such that the resulting graph does
not contain any tree on h + 1 vertices as a (not necessarily induced) subgraph, that is, we
delete at most k£ edges in order to obtain a graph in which every component contains at
most h vertices. This is desirable from the point of view of restricting the spread of a disease
in transmission networks. Enright and Meeks (Algorithmica, 2018) gave an algorithm to
solve Tp+1-FREE EDGE DELETION whose running time on an n-vertex graph G of treewidth
tw(G) is bounded by O((tw(G)h)*(@n). However, it remains open whether the problem
might belong to FPT when parameterized only by the treewidth tw(G); they conjectured
that treewidth alone is not enough, and that the problem is W[1]-hard with respect to this
parameterization. We resolve this conjecture by showing that 7,,1-FREE EDGE DELETION
is indeed W[1]-hard when parameterized by tw(G) alone. We resolve two additional open
questions posed by Enright and Meeks (Algorithmica, 2018) concerning the complexity of
Tni1-FREE EDGE DELETION on planar graphs and 7,,1-FREE ARC DELETION. We prove
that the 7,,1-FREE EDGE DELETION problem is NP-complete even when restricted to
planar graphs. We also show that the 7,,1-FREE ARC DELETION problem is W|2]|-hard
when parameterized by the solution size on directed acyclic graphs. This chapter is based
on the papers |66 [62].
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9.2 7T,.1-FREE EDGE DELETION parameterized by vertex

cover number

In this section, we present an FPT algorithm for the 7,,1;-FREE EDGE DELETION problem

parameterized by the vertex cover number. We prove the following theorem:

Theorem 9.2.1. 7,,1-FREE EDGE DELETION is FPT when parameterized by the vertex

cover number of the input graph.

To prove this theorem we formulate the problem as an Integer Linear Programming prob-
lem and use an algorithm of Fellows et al. [47] that solves parameterized minimization ILPs
in FPT time when parameterized by the number of variables. Without loss of generality
we assume that the graph has no isolated vertices. Let S be a vertex cover of G = (V, E)
of size k. We denote by I the independent set V' \ S. We partition the independent set
I into at most 2% twin classes Iy, I, ..., Isx, where some of them can also be empty. Two
vertices u and v are in the same twin class if N(u) = N(v). Our goal is to minimize the size
of E' C E(G) such that after deleting E’ from G, each connected component of the result-
ing graph has at most h vertices. First, we guess the intersection of S with the connected
components in G=0G \ E’. Tt is clear that the number of guesses is equal to the number
of different partitions of the k-element set S, which is equal to the Bell number By. It is

known that B, = 13 ’l—]: See [104]. For every guess, we will reduce our problem to an
i=0

integer linear progranzming (ILP) where the number of variables is a function of the vertex
cover number k. Since integer linear programming is fixed-parameter tractable when param-
eterized by the number of variables, we will conclude that our problem is fixed-parameter
tractable when parameterized by the vertex cover number. Let us consider a particular par-
tition P = {S1,5s,...,S¢} of S where ¢ < k. For a given partition P of S, we call an edge
a cross edge if both endpoints of that edge are in S but one endpoint in S; and the other
endpoint in S; such that ¢ # j. We denote the number of cross edges of partition P by cr(P).

ILP Formulation: Given a partition P = {51, 5s,...,S¢} of S, let C; be the component of
G such that SN C; = S;for 1 < i < (. Let Cpyq be the collection of size one components
in G such that SN Ciy1 = 0. For each I; and C}, we associate a variable x;; that indicates

|I; N C;| = x;;, that is, x;; denotes the number of vertices in twin class [; that go to Cj.
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Because the vertices in I; have the same neighbourhood, the variables z;; determine the
components uniquely and hence determine the required set of edges E' C F(G). We add

the following constraints to ILP. The vertices of each twin class I; is distributed among the

components C1,Cy, ..., Cp and Cyiq. Thus we have the following constraints:
+1
> wy =L forall 1<i<2b (9.1)
j=1

We want each connected component Cj in the resulting graph G to have at most h vertices.

Thus we have the following constraint:

2k

inj +15;| <h forall 1< </ (9.2)

i=1
Note that every vertex in I; has the same set of neighbours in S. Thus if a vertex v € I; goes
to C; then we have to remove all edges between v and S\ S}, so that Cy, Cs, ..., Cp and Cpyy
remains distinct components. Therefore, if x;; vertices of I; go to C;, then we need to remove
in total |[Ng\s,(v)| X x5 edges, where v is a vertex in /;. Hence we want to minimize the

following objective function:

2k p41

cr(P)+ > ) |Nsys, (vi)] x a5 (9.3)

i=1 j=1

where Sy 1 = ), cr(P) is the number of cross edges of partition P and v; is a vertex in
the twin class I;. In the following, we present an ILP formulation of the 7, ,;-FREE EDGE
DELETION problem, where a partition P = {S;,Ss,...,S,} of S is given:
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2k p41

Minimize cr(P)+ Y > [Nsys, (v7)] x x5
i=1 j=1

Subject to

0+1
inj =] forall 1<i< oF
j=1

2k

=1

In the formulation for 75, ;-FREE EDGE DELETION, we have at most 2¥(k + 1) variables.
The value of objective function is bounded by n? and the value of any variable in the integer
linear programming is bounded by n. The constraints can be represented using at most
O(2%logn) bits. Lemma implies that we can solve the problem with the guess P in
FPT time. There are at most B; choices for P, and the ILP formula for a guess can be
solved in FPT time. Thus Theorem [9.2.1] holds.

Now we make two important remarks:

Remark 9.2.1. We argue that the vertex cover number and the solution size are incompa-
rable. Let G be the disjoint union of ¥ cliques of size h > 2 each, and an arbitrary vertex
a of the first clique is adjacent to an arbitrary vertex b of the second clique. Clearly £
has to include only (a,b) so that each connected component in G'\ E’ has at most h vertex.
Thus the solution size is equal to 1, while the vertex cover number is (2 —1) =n — 3. On
the other hand consider the graph G = K ,_;. Clearly, the solution size is equal to n — h
while the vertex cover number is 1. As the vertex cover number and the solution size are
incomparable, the question of whether the 7,,;-FREE EDGE DELETION problem is FPT

parameterized by the solution size remains open.

Remark 9.2.2. Usually, algorithms for the vertex cover number can be adapted for twin
cover. Here we observe that our algorithm for the vertex cover number cannot be adapted
to twin cover. Let X be a twin cover of G of size k. Then G\ X is a disjoint collection of
cliques. We can partition G\ X into at most 2* twin classes, where some of them can also
be empty. Two cliques are in the same twin class if they have the same neighbourhood in
X. While designing an FPT algorithm parameterized by the vertex cover number, for each

I; and C}, we associate a variable z;; that indicates |I; N C;| = z;;, that is, z;; denotes the
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number of vertices in twin class I; that go to C;. Here as each twin class consists of cliques
of arbitrary sizes, we need to introduce a variable for each clique to denote the number of
vertices of the clique that go to the solution. As the number of cliques are arbitrary, the
number of variables would not be bounded by a function of k. Therefore, the question of
whether the 7, ,1-FREE EDGE DELETION problem is FPT parameterized by the twin cover

number remains open.

9.3 7,.1-FREE EDGE DELETION parameterized by treewidth

To show W|[1|-hardness of 7,.1-FREE EDGE DELETION, we reduce from MINIMUM MAXI-
MUM OUTDEGREE, which is known to be W[1|-hard parameterized by the treewidth of the
graph [123]. An orientation of an undirected graph is an assignment of a direction to each
of its edges. The MINIMUM MAXIMUM OUTDEGREE Problem (MMO) takes as input an
undirected, edge-weighted graph G = (V, E, w), where V| E, and w denote the set of vertices
of G, the set of edges of G, and an edge-weight function w : E — Z*, respectively, and asks
for an orientation of GG that minimizes the resulting maximum weighted outdegree taken over

all vertices in the oriented graph. More formally

MINIMUM MAXIMUM OUTDEGREE

Input: An undirected edge-weighted graph G = (V, E,w), where w denote an edge-
weight function w : E — Z* where the edge weights w are given in unary, and a positive
integer r.

Question: Is there an orientation of the edges of G such that, for each v € V(G), the

sum of the weights of outgoing edges from v is at most r?

Theorem 9.3.1. The T1-FREE EDGE DELETION problem is W/1]-hard when parameter-
1zed by the treewidth of the input graph.

Proof. Let I = (G = (V, E,w),r) be an instance of MINIMUM MAXIMUM OUTDEGREE. We
construct an instance I’ = (G’, k) of T,41-FREE EDGE DELETION the following way. See
Figure The construction of G’ starts with V(G') := V(G) and then add the following

new vertices and edges.
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1. For each edge (u,v) € E(G), create a set of w(u,v) vertices Vi, = {21",..., 230, }-
Make u and v adjacent to every vertex of V,,. For every 1 <i < w(u,v)— 1, introduce
an edge (zi", zi;) and an edge (zyy, ., 21").

2. Letw= > w(e)and h =4w. Foreachu € V(G), weaddaset V,, = {«¥},... 2} |}

e€E(Q)
of h —r — 1 new vertices and make them adjacent to w.

3. We set k£ = w.

Figure 9.1: The reduction from MINIMUM MAXIMUM OUTDEGREE to Tj,-FREE EDGE
DELETION in Theorem (a) An instance (G, r) of MINIMUM MAXIMUM OUTDEGREE
with » = 3. The orientation (a,d), (d,c), (¢,b), (b,a) satisfies the property that for each
v € V(G), the sum of the weights of outgoing edges from v is at most 3. (b) The graph G’
produced by the reduction algorithm.

Clearly I’ can be computed in time polynomial in the size of I. We now show that the
treewidth of G’ is bounded by a function of the treewidth of G. We do so by modifying an

optimal tree decomposition 7" of GG as follows:

e For every edge (u,v) of G, we take an arbitrary node in 7" whose bag X contains both
u and v; add to this node a chain of nodes 1,2,...,w(u,v) — 1 such that the bag of
node 1 is

uv UV UV
XU{xl 7131‘ 7Ii+1 .
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e For every edge u € V(G), we take an arbitrary node in 7" whose bag X contains u.
Add to this node a chain of nodes 1,2,...h — (r + 1) such that the bag of node i is
X U{z}!} where z} € V,.

It is easy to verify that the result is a valid tree decomposition of G’ and its width is at most
the treewidth of G plus three. Now we show that our reduction is correct. That is, we prove
that I = (G = (V, E,w),r) is a yes instance of MINIMUM MAXIMUM OUTDEGREE if and
only if I’ = (G', k) is a yes instance of T, 1-FREE EDGE DELETION. Let D be the directed
graph obtained by an orientation of the edges of GG such that for each vertex the sum of the

weights of outgoing edges is at most . We claim that the set of edges

E= |J {waz)|zeVi}CEG)

(u,w)EE(D)

is a solution of I’. In Figure the orientation (a,d), (d, ), (¢, b), (b, a) satisfies the prop-
erty that for each v € V(G), the sum of the weights of outgoing edges from v is at most
3. Therefore E(D) = {(a,d), (d,c), (c,b),(b,a)} and E' = {(d,x) | x € Vgt U{(d,z) | z €
Vi tU{(b,z) | © € Ve }U{(a,x) | © € Vg }. Note that the edges of D are directed. Clearly, we
have | E'| = w. We need to show that G =G \ £’ does not contain any connected components
of size h+1. Observe that every connected component in G’ contains exactly one vertex from
V(G). For each u € V(G') N V(G), let C, be the component of G’ that contains u. Then

Cu={utuV,u U Vi Foreachu € V(D), let wy ; denote the sum of the weights of
(uv)€E(D)

outgoing edges of vertex w in D. Note that for every u € V(G), ‘ U Vuv‘ =wip ST
(u,v)EE(D)
and |V,| = h — (r +1). Therefore we have |C,| <1+ h—(r+1)+r=h.

For the reverse direction, let £/ C E(G") be a solution for I’, that is, |E'| = w and G"\ £’
does not contain any connected component of size more than h. We first claim that deletion
of E' from G’ destroys all paths between any pair of vertices u,v € V(G) N V(G’). For the
sake of contradiction, let us assume that there is a path between u and v in G = \ E.
Note that u (resp. v) is adjacent to h — (r 4 1) vertices of V,, (resp. V,,) in G’. If there is a
path between v and v in G"\ £’ then we get a connected component C,,, consists of u, v and
at least |V, |+|V,| —w vertices of V,,UV,. The reason is this. If £’ contains s edges between u

and V,, or between v and V,,, then C,,, contains |V, |+|V,|—s vertices of V,,UV,. Thus, we have
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|Cuo| > 2h —2(r+1) —w+2
=8w—2r—w
> 8w — 2w — w as r < w
= dw
>4w+1
=h+1

This contradict the assumption that G’ \ E’ does not contain any connected component of
size more than h. This concludes the proof of the claim.

As we delete at most w edges, a solution £’ must contain either F,, = {(u, x) |z € Vuv}
or By, = {(v,z) | x € V,,} for every edge (u,v) € E(G); otherwise there will be a path
between u and v. We now define a directed graph D by V(D) = V(G) and

E(D) = {(u,v) | B € B} J{(v,0) | B C F'}.

We claim that for each vertex x in D the sum of the weights of outgoing edges is at most
r. For the sake of contradiction, suppose there is a vertex x in D for which wiye > 1 In
this case, we observe that z is adjacent to h — (r + 1) +wg > h—(r+1)+ (r+1)=h
vertices in graph G’ = G’ \ E’. This is a contradiction as vertex x and its h neighbours form

a connected component of size at least h + 1, which is a forbidden graph in G O

9.4 7,.1-FREE EDGE DELETION on planar graphs

Enright and Meeks [44] have discussed the importance of studying 7,1-FREE EDGE DELE-
TION on planar graphs. We show that 7;,,1-FREE EDGE DELETION remains NP-complete
even when restricted to planar graphs. To prove this, we give a polynomial time reduction
from MULTITERMINAL CUT. The MULTITERMINAL CUT problem can be defined as follows:
Given a graph G = (V, E), aset T = {t1,t, ..., t,} of p specified vertices or terminals, and a
positive weight w(e) for each edge e € F, find a minimum weight set of edges E' C E such
that the removal of £’ from F disconnects each terminal from all the others. Dahlhaus et

al. [32] proved the following result:
Theorem 9.4.1. [32] If p is not fized, the MULTITERMINAL CUT problem for planar graphs
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1s NP-hard even if all edge weights are equal to 1.

Theorem 9.4.2. The T,.1-FREE EDGE DELETION problem is NP-complete even when re-

stricted to planar graphs.

Proof. It is easy to see that the problem is in NP. In order to obtain the NP-hardness result
for the 7,.1-FREE EDGE DELETION problem, we obtain a polynomial reduction from the
MUTLITERMINAL CUT problem on planar graphs with all edge weights equal to 1. Let
I = (G, T = {t1,ta,...,t,},¢) be an instance of MULTITERMINAL CUT. The objective in
MULTITERMINAL CUT is to find a set £’ C E of at most ¢ edges such that the removal of
E’ from E disconnects each terminal from all the others. We produce an equivalent instance
I' = (G',k,h) of Thy1-FREE EDGE DELETION in the following way. Start with G = G’
and then add the following new vertices and edges. For each ¢t € T, we introduce a set
V; of % + ¢ vertices and make them adjacent to t. We take h = 100n3. This completes

the construction of G'. We set k = £. Let us now show that I and I’ are equivalent instances.

Assume first that there exists a set £/ C E(G) of at most ¢ edges such that the removal
of E' from E disconnects each terminal from all the others. We claim that the same set
E' C E(G') is a solution of I’. That is, we show that

G =G\FE

does not contain any connected component of size h + 1. For each t € T, let C; be the
component of G’ that contains . Note that ¢ is adjacent to every vertex in V; and some
vertices in V(G). Therefore the size of Cy is at most n + % + ¢. This is true because
there is no path between t and any vertex in Vp, U {t'} for all ' € T', t # t'. Thus, we have
Gy < n+ —100”23+ Ly

<n+ —100”23 1B

<t 100n® +1 N (n)

- 2 2

< 100n*

=h
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Hence the size of each component in G’ is at most h.

Conversely, suppose that there exists a set £’ C E(G’) of k edges such that G = \ £
does not contain any connected component of size h + 1. We claim that there is no path
between ¢; and ¢; in G forall 1 < 1,7 < k and 7 # j. For the sake of contradiction, assume
that there is a path between terminals ¢; and ¢; in G'. Note that t; and t; are each adjacent
to % + ¢ many pendent vertices in GG/, and we have deleted at most & = ¢ many edges.
Therefore the connected component containing ¢; and t; contains at least h + 1 vertices,
which is a contradiction. This concludes the proof of the claim.

We now claim S = E' N E(G) is a solution of I. That is, we claim that G \ S disconnects
each terminal from all the others. Note that |S| < |E'| < ¢. For the sake of contradiction,
assume that there is a path between two terminals ¢; and ¢; in G \ S. Then there is also a
path between t; and ¢; in G’ \ E’. Note that if there exists a path between two terminals ¢;
and ¢; in G' \ £ then clearly we get a connected components of size h 4 1, a contradiction.
Therefore G\ S disconnects each terminal from all the other terminals, and hence [ is a

yes-instance. N

9.5 7,.1-FREE ARC DELETION parameterized by solution

size

Enright and Meeks |44] explained the importance of studying 7,,1-FREE ARC DELETION.
A directed acyclic graph (DAG) is a directed graph with no directed cycles. One natural
problem mentioned in [44] is to consider whether there exists an efficient algorithm to solve
this problem on directed acyclic graphs. In this section, we show that the problem is W|2|-
hard parameterized by the solution size k, even when restricted to directed acyclic graphs
(DAG). We prove this result via a reduction from HITTING SET. In the HITTING SET
problem we are given as input a family F over a universe U, together with an integer k&, and
the objective is to determine whether there is a set B C U of size at most k such that B has
nonempty intersection with all sets in F. It is proved in [31] (Theorem 13.28) that HITTING
SET problem is W|2|-hard parameterized by the solution size.

Theorem 9.5.1. The T,.1-FREE ARC DELETION problem is W[2/-hard parameterized by
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Figure 9.2: The graph in the proof of Theorem constructed from HITTING SET instance
U= {3717 T2, x37$4}7 F = {{xlwx?}a {x% Qfg}, {x37$4}} and k = 2.

the solution size k, even when restricted to directed acyclic graphs.

Proof. Let I = (U, F,k) be an instance of HITTING SET where U = {z1,29,...,2,}. We
create an instance I’ = (G, k', h) of T, .1-FREE ARC DELETION the following way. For every
x € U, create two vertices v, and v/, and add a directed edge (v,,v)). For every F' € F,
create one vertex vp. Next, we add a directed edge (vp,v,) if and only if x € F. For each
x € U, we add a set V, of % many new vertices and add a directed edge from v/, to every
vertex of V.. We specify the value of h at the end of the construction. For each vertex

F € F, we add a set Vg of (h+ 1) — > |V,| new vertices and add a directed edge from vp
zeF
to every vertex of Vp. Finally, we set ¥’ = k and h = n° for some large constant c¢. This

completes the construction of G'. Next, we show that I and I’ are equivalent instances.

Let us assume that there exists a subset S C U such that |S| < k and SN F # () for all

F € F. We claim that every vertex in G/ = G’ \ U (vg,v.) can reach at most h vertices.
zeS

Let us assume that there exists a vertex in G’ which can reach more than h vertices. Clearly
that vertex must be vp for some ' € F. Without loss of generality assume that z; € SN F.

As we have removed the edge (vg,,v},) from G, clearly vp cannot reach any vertex in V.

203



Note that in such a case vp cannot reach more than h vertices as h = n® for some large

constant. In particular, vp can reach at most h+1— (> [Vo|)+( > |Vi|) < h vertices.
zeF zeF\{z1}

In the other direction, let us assume that there exists a set ' C F(G’) such that |E'| < k

and every vertex in G' = G’ \ £’ can reach at most h vertices. First we show that, given a

solution E’ we can construct another solution E” such that £” C J (v,,v)) and |E”| < |E'|.
zeU
To do this, we observe that the only vertices that can possibly reach more than h vertices

are vp. Note that if £ contains an edge of the form (vp,u) for some u € Vi then we can
replace it by an arbitrary edge (v,,v,) for some x € F. This will allow us to disconnect at
least % vertices from vp rather than just 1. Similar observation can be made for edges of

type (vp,v,) for some x € F by replacing it with edge (v,,v)). Therefore, we can assume

that E” C |J (vs,v,). Next, we show that if there exists a vertex vg such that for every
zeU
x € F we have (v;,v),) ¢ E” then vp can reach h + 1 vertices. Clearly, vg can reach Vg,

{v, | x € F} and also {V,, | x € F'}. Due to construction, this set is of size more than h. This
implies that for every F' € F, there exists an edge (v,,v},) for some x € F' which is included
in £”. As |E"| < k, we can define S = {z | (v,,v,) € E"}. Due to earlier observations, S is

a hitting set of size at most k. O

9.6 Closing Remarks and Future Directions

The main contributions is that the 754 ;-FREE EDGE DELETION problem is W|1|-hard when
parameterized by the treewidth of the input graph. Thus we resolved a conjecture stated
by Enright and Meeks [44] concerning the complexity of 7T, +1-FREE EDGE DELETION pa-
rameterized by the treewidth of the input graph. We also studied the following important
open questions stated in [44]: The 7p41-FREE EDGE DELETION problem is NP-complete
even when restricted to planar graphs; and the 7,,1-FREE ARC DELETION problem is
W]{2]-hard parameterized by the solution size k, even when restricted to directed acyclic
graphs. However, it remains open whether 7,,;-FREE EDGE DELETION problem is FPT
when parameterized by the solution size k. See Figure for a schematic representation
of the relationship between selected graph parameters [94]. Note that A — B means that
there exists a function f such that for all graphs, f(A(G)) > B(G); therefore the exis-

tence of an FPT algorithm parameterized by B implies the existence of an FPT algorithm
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(\\VC//

Figure 9.3: Relationship between vertex cover (vc), neighbourhood diversity (nd), twin cover
(tc), modular width (mw), feedback vertex set (fvs), pathwidth (pw), treewidth (tw) and
clique width (cw). Arrow indicate generalizations, for example, treewidth generalizes both
feedback vertex set and pathwidth.

parameterized by A, and conversely, any negative result parameterized by A implies the
same negative result parameterized by B. Gaikwad and Maity [61] proved that the Tp1-
FREE EDGE DELETION problem is fixed-parameter tractable when parameterized by the
vertex cover number of the input graph. Here we have proved that the 7;,.1-FREE EDGE
DELETION problem is W[1]-hard when parameterized by the treewidth of the input graph.
The parameterized complexity of the 7;,.1-FREE EDGE DELETION problem remains open
when parameterized by other structural parameters such as feedback vertex set, pathwidth,

treedepth, neighbourhood diversity, cluster vertex deletion set, modular width etc.
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Chapter 10

MaxMin Separation Problems

In this chapter, we study the parameterized complexity of the MAXMIN versions of two fun-
damental separation problems: MAXIMUM MINIMAL st-SEPARATOR and MAXIMUM MINI-
MAL ODD CYCLE TRANSVERSAL (OCT), both parameterized by the solution size. In the
MAXIMUM MINIMAL st-SEPARATOR problem, given a graph G, two distinct vertices s and ¢
and a positive integer k, the goal is to determine whether there exists a minimal st-separator
in G of size at least k. Similarly, the MAXIMUM MINIMAL OCT problem seeks to deter-
mine if there exists a minimal set of vertices whose deletion results in a bipartite graph, and
whose size is at least k. We demonstrate that both problems are fixed-parameter tractable
parameterized by k. Our FPT algorithm for MAXIMUM MINIMAL st-SEPARATOR answers
the open question by Hanaka, Bodlaender, van der Zanden & Ono [TCS 2019].

In contrast to MAXMIN variants of several other deletion problems on graphs, where
proving the existence of an FPT algorithm is not very difficult and treewidth-based win-win
approaches usually work, the problems considered in this work pose great difficulties even
if one does not care about the explicit running times of the FPT algorithm. We showcase
several barriers that both these problems pose and show how a clever combination of different

combinatorial ideas finally work in overcoming them.

One unique insight from this work is the following. We use the meta-result of Loksh-
tanov, Ramanujan, Saurabh & Zehavi [ICALP 2018| that enables us to reduce our problems
to highly unbreakable graphs. This is interesting, as an explicit use of the recursive under-

standing and randomized contractions framework of Chitnis, Cygan, Hajiaghayi, Pilipczuk
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& Pilipczuk [SICOMP 2016| to reduce to the highly unbreakable graphs setting (which is
the result that Lokshtanov et al. tries to abstract out in their meta-theorem) does not seem

obvious because certain “extension” variants of our problems are W|1|-hard.

Another interesting feature of these results is that in the MAXMIN setting, it is not
straight-forward to use an FPT algorithm for the st-SEPARATOR problem to design an FPT
algorithm for the OCT problem (unlike in many other settings). In fact, our FPT algorithms
for both the problems are independent of each other. This chapter is based on the paper
[59].

10.1 Challenges and Our Approach

Problem 1: MaxMin st-Sep. In this work, for the first time, we use the power of highly
unbreakable instances to show fixed-parameter tractability of some MAXMIN separation
problems. For two positive integers ¢, k, a graph G is called (g, k)-unbreakable if no vertex
set of size at most k can disconnect two (large) sets of size at least ¢ each. For the purposes
of an informal discussion, we say a graph is unbreakable if it is (g, k)-unbreakable for some

values of ¢ and k.

Chitnis et al. [28] developed a win-win approach based on this unbreakable structure of the
graph. The approach has two parts: recursive understanding and randomized contractions.
In the first part, a large enough part of the input graph is detected that is unbreakable and
has small boundary to the rest of the graph. In the second part, a family of “partial solutions”
are computed for this unbreakable part of the graph depending on how the solution for the
whole graph interacts with its boundary. If the unbreakable part is large enough, then there
exists an irrelevant edge/vertex that does not participate in the computed partial solutions,

which helps in reducing the size of the graph.

Unfortunately, at the first glance it looks impossible to use this approach for solving
any of the two problems MAXMIN st-SEP and MAXMIN OCT. The problem is that in the
above approach one needs to find partial solutions on unbreakable graphs for a more general
“extension-kind” of problem. In particular, the family of partial solutions should be such
that if there exists a solution for the whole graph, then one should be able to replace the

part of this solution that intersects with the unbreakable part, with one of the computed
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partial solutions. To do so, for example, it seems necessary to, in some implicit way at
least, guess how an optimum solution of the whole graph intersects with the boundary of
this unbreakable part and the partial solution should at least try to be “compatible” with
this guess. The bottleneck here is that given a vertex v, finding whether the graph G has
any minimal st-separator containing v is NP-hard (see Section . Thus, the problem
of determining, given a subset of vertices X, whether G has a minimal st-separator of size
at least k, that contains X, is W[l]-hard (it is in fact para-NP-hard) (see Section [10.3).
Therefore, a first glance suggests that computing “partial solutions” may be W[1|-hard in
general. One can ask if the hardness holds even when the graph is unbreakable (which is our
scenario). It turns out yes, because a result by Lokshtanov et al. [103], shows that the FPT
algorithm for unbreakable graphs can be lifted to an FPT algorithm on general graphs for
problems definable in Counting Monadic Second Order (CMSO) Logic. Since the extension
version is also CMSO definable, there should not be an FPT algorithm for the extension

version even on unbreakable graphs.

Despite this issue, we show that the core lies in solving the problem on unbreakable
graphs, and in fact the problem on unbreakable graphs is FPT using non-trivial insights. In
fact the result of Lokshtanov et al. [103] comes to rescue. In [103] Lokshtanov et al. show that,
in order to show fixed-parameter tractability of CMSO definable problems parameterized by
the solution size (say k), it is enough to design FPT algorithms for such problems when
the input graph is (g, k)-unbreakable, for some large enough ¢ that depends only on k.
The highlight of this result, compared to explicitly doing the above-mentioned approach
of recursive understanding and randomized contractions is that, this results says that it is
enough to solve the same problem on unbreakable graphs. This allows us to skip taking the

provably hard route of dealing with extension-kind problems.

After overcoming the above issues, we can safely say that the core lies in designing an
FPT algorithm for the unbreakable case, which itself is far from obvious. We give a technical
overview of this phase in Section [10.2]

Problem 2: MAXMIN OCT. The challenges continue for the MAXMIN OCT prob-
lem. In parameterized complexity, the first FPT algorithm for the classical ODD CYCLE
TRANSVERSAL (OCT) problem parameterized by the solution size, introduced the tech-
nique of Iterative Compression [115]. Using this, it was shown that OCT reduces to solving

at most 3* - n instances of the polynomial-time st-SEPARATOR problem. Most algorithms
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for different variants of the OCT problem, like INDEPENDENT OCT, use the same frame-
work and reduce to essentially solving the variant of st-SEPARATOR, like INDEPENDENT
st-SEPARATOR [102].

For the MAXMIN variant, unfortunately this is not the case. In fact our FPT algorithm
for MAXMIN OCT is independent of our FPT algorithm for MAXMIN st-SEP. The reason
is again that finding a minimal odd cycle transversal set (oct) that extends a given vertex
is NP-hard (see Section . At the core of the Iterative Compression based approach for
OCT, a subset of vertices X is guessed to be in the solution, and after deleting X, the
problem reduces to finding an st-separator, for some s,t. In particular, the final solution is
this set X union a minimum st-separator. For the MAXMIN case, this amounts to finding a
minimal st-separator that together with X forms a minimal oct. Since the extension version
of both the MAXMIN st-SEPARATOR and MAXMIN OCT are para-NP-hard, this leaves
little hope to use the same approach for MAXMIN versions.

Having eliminated this approach, we again go back to the approach via unbreakable
graphs. This time again the core lies in designing an FPT algorithm when the graph is
highly unbreakable and this algorithm require lot more insights than that of the MAXMIN
st-SEP. We give a technical overview of this stage in Section[10.2]

10.1.1 The Sunflower Lemma

Now, we state the Sunflower Lemma. We first define the terminology used in the statement
of the next lemma. Given a set system (U, F), where U is a set and F is a family con-
taining distinct subsets of U, a sunflower with k petals and a core Y is a collection of sets
S1,52,...,5, € F such that S;NS; =Y for all i # j. The sets S; \ Y are called the petals
of this sunflower. If the sets in F are distinct and k > 2, then none of the petals of the
sunflower are empty. Note that a family of pairwise disjoint sets is a sunflower (with an

empty core).

Theorem 10.1.1 (Sunflower Lemma, [45] 31]). Let F be a family of distinct sets over a
universe U, such that each set in F has cardinality exactly d. If |F| > d!(k — 1)¢, then F

contains a sunflower with k petals and such a sunflower can be computed in time polynomial

in |F|, |U|, and k.
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10.2 Owur Results and Technical Overview

Below we state two main theorems of this work.
Theorem 10.2.1. MAXIMUM MINIMAL st-SEPARATOR parameterized by k is FPT.

Theorem 10.2.2. MAXIMUM MINIMAL OCT parameterized by k is FPT.

As discussed earlier, to prove both our theorems we first show that both MAXMIN st-
SEP and MAXMIN OCT are CMSO definable. We then use Proposition |10.2.3] to reduce to

solving the problem on unbreakable graphs.

Theorem 10.2.3 (Theorem 1, [103]). Let ¢ be a CMSO formula. For all ¢ € N, there
exists s € N such that if there exists an algorithm that solves CMSOfp] on (s, c)-unbreakable
structures in time O(n?) for some d > 4, then there exists an algorithm that solves CMSOp)|

on general structures in time O(n?).

In particular, to prove Theorems [10.2.1] and [10.2.2] it is enough to prove Theorems [10.2.4
and [10.2.5

Theorem 10.2.4. For positive integers q,k > 1, MAXIMUM MINIMAL st-SEPARATOR on

(q, k)-unbreakable graphs on n wvertices can be solved in time (k — 1)24 . n®W),

Theorem 10.2.5. For any positive integers q, k > 1 MAXIMUM MINIMAL OCT on (¢, k)-
unbreakable graphs is FPT parameterized by q + k.

MAXMIN st-SEP on (¢, k)-unbreakable graphs. To prove Theorem we develop a
branching algorithm which exploits the unbreakability of the input graph. The key observa-
tion behind the algorithm is that for any two vertex sets S and T such that s € S, ¢t € T and
G[S] and G[T] are connected, every minimal ST-separator is also a minimal st-separator. As
we are working on (g, k)-unbreakable graphs, if we manage to find such sets where |S| > ¢
and |T| > ¢, then every minimal ST-separator has size at least k. In this case, we can
construct a minimal S7T-separator greedily in polynomial time, which can then be returned

as a solution. Therefore, the goal of our branching algorithm is to construct such sets S and
T.

The algorithm begins with S = {s} and T' = {t}. We use a reduction rule which ensures
that at any point N(S) and N(7T') (the neighborhoods of S and 7T") are minimal ST -separators
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in G. Clearly, we can assume that |N(S)| < k and |N(T)| < k; otherwise, we have already
found a solution. A crucial observation is that if there exists a minimal ST-separator Z of
size at least k, then there exists a vertex u € N(S)\ N(T) (resp. u € N(T) \ N(5)) such
that u ¢ Z. This implies that such a vertex u remains reachable from S even after removing
the solution Z. This observation allows us to grow the set S to SU{u} (resp. 7' to T'U{u}).
Since |[N(S)| < k (resp. |N(T)| < k), one can branch on all possible vertices u € N(S5)
(resp. w € N(T')) and in each branch grow S (resp. T'). Based on whether min(|S|, |T) is | 5]
or |T|, we branch on the vertices in N(S)\ N(T) or N(T') \ N(S5), respectively, to increase
the size of these sets. Once both S and T have sizes of at least ¢, we can greedily construct

and output a minimal ST-separator (which is also a minimal st-separator) of size at least k.

MAXMIN OCT on (¢, k)-unbreakable graphs. The algorithm for this case uses two key
lemmas, both of which provide sufficient conditions for a yes instance. Here the input is a

(q, 2k)-unbreakable graph.

Our first key lemma (Lemma says that if there exists an induced odd cycle of
length at least 2¢ 4+ 2 in G, then there always exist a minimal oct in G of size at least k.
The proof of this result is based on a branching algorithm, which works similarly to the
branching algorithm for the MAXMIN st-SEPARATOR problem on (g, k)-unbreakable graphs,
by carefully selecting the sets S and T at the beginning of the algorithm. Note that we
cannot use this lemma, on its own as a a stopping criterion, because one does not know how
to find a long induced odd length cycle efficiently. Nevertheless, as we see later it becomes

useful together with our second sufficient condition.

Our second key lemma (Lemma, which states a second sufficient condition, says
that if there exists a large enough family of distinct (and not necessarily disjoint) induced
odd cycles in G of lengths at most 2¢g+ 1, then there always exists a minimal oct in G of size
at least k. The proof of this result relies on the Sunflower Lemma [45] 31], along with the
observation that the subgraph induced by the core of the sunflower must be bipartite. Such
a large sunflower then serves as a certificate that any oct which is disjoint from the core of
the sunflower is large. Also since the core is bipartite, there exists an oct that is disjoint

from it.

Another simple, yet important observation is that, if a vertex is not part of any induced

odd cycle, then deleting this vertex from the graph does not affect the solution. Again, the
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problem here is that determining whether a vertex passes through an induced odd cycle is

NP-hard (see Section [10.6), therefore we cannot use it as a reduction rule.

Finally, using these two sufficient conditions and the observation above, we provide an
FPT algorithm that, given a vertex x, either outputs an induced odd cycle containing x if it
exists, or concludes correctly that one of the two scenarios mentioned above occurs. In the
later situation we are done. Also if the induced odd cycle containing z, which is returned,
is long, then also we are done because of the first sufficient condition. If the algorithm
outputs, that there is no induced odd cycle through x, then we can safely delete x from the
graph and reduce its size. Because of deleting such vertices, the new graph may no longer
be unbreakable, in which case we start solving the problem on the reduced graph completely

from scratch.

By running this algorithm for each z € V(G), we reduce the graph to one where each
vertex is contained in some small induced odd cycle. If such a graph contains a sufficiently
large number of vertices, it guarantees the existence of a large family of distinct small induced
odd cycles in GG, each of length at most 2¢ + 2, in which case we can return a yes instance
because of the second sufficient condition. This result finally allows us to bound the number
of vertices in the graph by (¢gk)°@. We can then solve the problem using a brute-force

algorithm on this graph.

10.3 Para-NP-hardness for extending a vertex to a min-

imal st-separator

We begin by providing a characterization for vertices that can be a part of a minimal st-

separator.

Lemma 10.3.1. Let G be a graph containing vertices s and t. A vertexv € V(G) is in some

minimal st-separator Z if and only if there is an induced path between s and t containing v.

Proof. For the first part, assume there exists a minimal st-separator Z that contains v. By
the minimality of Z, there must be a path P between s and ¢ in the graph G — (Z \ {v}),
which passes through v. In other words, path P is such that no vertex in Z other than

v appears on it. However, since there is no induced path between s and ¢ through v, two
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vertices, say a and b, on P must be adjacent, with a lying between s and v, and b lying
between v and t. This creates a new path between s and ¢ that does not include any vertex

from Z, contradicting the assumption that Z is an st-separator.

For the second part, if there exists an induced path between s and t passing through v,
we can construct a minimal st-separator Z that includes v. According to Definition[10.4.1]
let S be the set of all vertices on the induced path from s to a, and T the set of all vertices
on the induced path from b to ¢, where a is a predecessor of v and b a successor of v. These
sets, S and T, serve as a certificate for the st-separator minimality for v. By Lemma

we can then construct a minimal st-separator that contains v. O

Lemma 10.3.2 (|80]). Given a graph G and any three arbitrary vertices s,t and v, deter-

maining if there exist an induced path between s and t through v is NP-hard.

Lemma (10.3.1)and Lemma [10.3.2| together shows that given a vertex v, it is NP-hard to

determine if there is a minimal st-separator containing v.

10.4 MAXIMUM MINIMAL st-SEPARATOR parameterized

by the solution size

The goal of this section is to prove Theorem [10.2.1

Let (G, k) be an instance of MAXMIN st-SEPARATOR. The goal is to reduce the task to de-
signing an algorithm for (¢, k)-unbreakable graphs. For this we first show that the problem
can be expressed in CMSO (in fact in MSO). Since MAXMIN st-SEPARATOR is a maximiza-
tion problem, the size of the solution can potentially be as large as O(n). To formulate a
CMSO sentence that is bounded by a function of k, we focus on a k-sized subset of the solu-
tion and encode the minimality of each of its vertices in a way that allows for its extension

to a “full-blown” minimal solution.

Lemma 10.4.1. MAXIMUM MINIMAL st-SEPARATOR 1is CMSO-definable with a formula of
length O(k).

Proof. The instance (G, k) is a yes instance of MAXMIN st-SEP if there exists Z C V(G) of
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size at least k such that G — Z has no st-path (equivalently s and ¢ are in different connected
components of G — Z), and for each v € Z, G — (Z \ {v}) has an st-path (that is s and ¢ are

in the same connected component of G — (Z \ {v})).

Alternately, suppose Z C V(G) (of arbitrarily large size) such that G — Z has no st-path,
and Z contains k distinct vertices vy, ..., vy such that for each i € [k], G—(Z\ {v;}) contains
an st-path. Then Z may not be a minimal st-separator but it always contains a minimal
st-separator of size at least k. In fact, (G, k) is a yes instance if and only if such a set Z
exists. These properties of Z can be incorporated as a CMSO formula 1) as follows, where

conn(U) is a CMSO sentence that checks whether a vertex set U induces a connected graph.
The CMSO description of CMSO can be, for example, found in [31].

szlZQV(G)(Elvl,vz,...,vkeZ( /\ Ui#yj.)

ATUCV(G)\ Z((s cU)A(teU)A conn(U))

k

/\—E]U e V(G)\(Z\{UZ}><(S eU)AN(t e U)/\conn(U)))

=1

It is clear that the size of the above formula v depends linearly on k. n

From Lemma [10.4.1| and Proposition [10.2.3] to prove Theorem [10.2.1} it is enough to
prove Theorem [10.2.4

Theorem (Theorem|10.2.4). For positive integers g,k > 1, MAXIMUM MINIMAL $t-SEPARATOR

on (q, k)-unbreakable graphs on n vertices can be solved in time (k — 1)%.n°0).

We prove Theorem [10.2.4]in Section As mentioned earlier, given a vertex set V', it
may not always be possible to extend it to a minimal st-separator. Below, we give a definition
for a certificate for the st-separator minimality of a set V' C V(G), which guarantees the

existence of a minimal st-separator that contains (extends) the set V.

Definition 10.4.1. Let G be a graph, s,t € V(G) and V' C V(G). We say that two sets of

vertices S and T serve as a certificate for the st-separator minimality for V' if the following
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conditions hold: s € S andt € T, SNT = 0, G[S] and G[T| are connected subgraphs,
Ec(S,T) =0, and for every v € V', the subgraph G[S UT U {v}] is connected. Note that
V'n(suT) = 0.

Lemma 10.4.2. Let G be a graph, and let s,t € V(G). If there exists a certificate for the
st-separator minimality of V' C V(G), then there ezists a minimal st-separator in G that

includes all the vertices of V.

Proof. Let S and T serve as a certificate for the st-separator minimality of V’. We will
construct a set V' C Z C V(G) \ (SUT) which is a minimal st-separator in G. The set Z
is constructed iteratively. Initialize Z := () and G’ := G[S UT]. Fix an arbitrary ordering of
the vertices in V(G) \ (SUT).

For each vertex v in the prescribed order:

e if G[SUT U{v}]is connected, then update Z := Z U {v};

e if G[SUT U {v}| is not connected, then update G’ := G[V(G’') U {v}], update S to
be the vertices reachable from old S in G’, and update T" to be the vertices reachable
from old T in G'.

The process continues until all vertices in V(G) \ V(G’) have been processed. The final
set Z is then returned as a minimal st-separator of G. Note that if the initial sets S and
T served as a certificate for the minimality of the st-separator V', then V' C Z, as the
subgraph G[S U T U {v}] will always be connected for each v € V' during every stage of the

above process. O

10.5 MAXIMUM MINIMAL st-Separator on (g, k)-unbreakable
graphs
In this section, we prove Theorem To prove Theorem [10.2.4] we design a branching

algorithm that maintains a tuple (G, S, T, k, q) where G is a (q, k)-unbreakable graph, S, T C
V(G) and ¢, k are positive integers. Additionally the sets S, T  satisfy the following properties.
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1. se SandteT,
2. SNT =10,
3. both G[S] and G[T] are connected and

4. BE(S,T)=0.

An instance (G, S, T, k, q) satisfying the above properties is called a valid instance. Given
a valid instance, we design a branching algorithm that outputs a minimal ST-separator of
G, which is disjoint from S UT, and has size at least k, if it exists. The algorithm initializes
S :={s} and T := {t}. Note that the above-mentioned properties of the sets S, T’ ensure that

at each stage of the algorithm, every minimal ST -separator is also a minimal st-separator.

The algorithm has one reduction rule (Reduction Rule, four stopping criteria (Reduc-
tion Rules and [5) and one branching rule (Branching Rule . The branching rule
is applied when neither the reduction rule nor the three stopping criterion can be applied.
The overall idea is the following. Observe that N(S) N N(T') is a part of any minimal S7-
separator. Therefore, if |[N(S) N N(T)| > k, then we can correctly report that G has a
minimal ST-separator of size at least k. Reduction Rule ensures that N(S) (resp. N(T'))
is a minimal ST-separator. Therefore when Reduction Rule |3|is no longer applicable, if
IN(S)| > k (resp. |N(T)| > k), then we can correctly report that G has a minimal ST-
separator of size at least k. In fact, |N(S)| (resp. |N(T')|) is a minimal ST-separator of
size at least k. Otherwise, we have that both |N(S)| < k and |N(T)| < k. In this case,
we use the branching rule. Say, without loss of generality that |[S| < |T'|. Since N(S) is a
minimal ST-separator, but its size is strictly less than £ and every minimal ST-separator
contains N(S) N N(T'), there exists a vertex in N(S) \ N(T') that does not belong to the
solution (if there exists a solution of size at least k). In this case we branch on the vertices
of N(S)\ N(T'). If we guess that a vertex v € N(S) does not belong to the solution, since
v € N(5), v remains reachable from S after removing the solution. In this case, we update
S := S U {v}. Therefore, each application of the branching rule increases the size of the
smaller of the two sets S or T. When both |S| > g and |T'| > ¢, from the (g, k)-unbreakability
of G, we know that every ST-separator of G has size at least k. And hence there is a minimal

st-separator of size at least k.
Below we formalize the above arguments. Given I = (G, S, T\ k, q), we define its measure
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wu(I) = qg—min(|S],|T|). We state the reduction rules and a branching rule below. We apply

the reduction rules in order exhaustively before applying the branching rule.

Lemma 10.5.1. Let I = (G, S,T,k,q) where G is (q,k)-unbreakable. If u(I) < 0, then

every minimal ST'-separator, which is disjoint from S UT, is of size at least k.

Proof. If (1) < 0, then ¢ < min(|S|,|T"|). For the sake of contradiction, let us assume
that there exists a minimal S7T-separator Z in GG, which is disjoint from S UT', and has size
strictly less than k. Note that G \ Z contains two connected components of size at least
q each: one containing S, say Cg, and the other containing 7. Consider the separation
(CsU Z,V(G)\ Cs) of G. This is a witnessing separation that G is (g, k)-breakable, which

is a contradiction. O

The safeness of Reduction Rule[1]is immediate from Lemma[10.5.1]
Reduction Rule Max Min st-sep 1. If u(I) <0, then report a yes instance.
Reduction Rule Max Min st-sep 2. If |[N(S) N N(T)| > k, then report a yes instance.

Lemma 10.5.2. Reduction Rule[Zis safe.

Proof. The above reduction rule is safe because the sets S and T serve as a certificate for
the st-separator minimality of the vertex set N(S)NN(T'). From Lemma|10.4.2| there exists
a minimal st-separator in G that contains N(S) N N(T'). Since |N(S) N N(T)| > k, we

conclude that G contains a minimal st-separator of size at least k. n

Lemma 10.5.3. If there exists v € N(S) (resp. v € N(T')), such that every path from v to
any vertex of T (resp. S) intersects N(S) \ {v} (resp. N(T')\{v}), or there is no path from
v to any vertex of T (resp. S), then there is no minimal ST-separator which is disjoint from
SUT and that contains v.

Proof. When v has no path to any vertex of T, then such a vertex cannot lie on any S7T-path
and hence, is not a part of any minimal S7T-separator.

Suppose now that v € N(S) and every path from v to any vertex of T" intersects N(S) \
{v}. The other case when v € N(T') is symmetric. For the sake of contradiction, say there
exists a minimal ST-separator Z such that v € Z and ZN(SUT) = (). This implies that in
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the graph G — Z, there is no path from any vertex in S to any vertex in 7', but in the graph
G — (Z \ {v}), such a path, say P, exists. Let P be a path from s’ to ¢ in G — (Z \ {v}),
where s’ € Sand t/ € T.

Since v cannot reach any vertex of 7' (in particular ¢') in G, without traversing another
vertex, say u, in N(S), consider the u to ¢’ subpath of P (which does not contain v). Since
u € N(S), let " € N(u)nS. Since ZN (SUT) =, there exists a path P’ from s” to ' in
G — (Z\ {v}) that does not contain v (take the edge (s”,u), followed by the u to t' subpath
of P). This contradicts that Z is an ST-separator. ]

The following reduction rule ensures that both N(.S) and N(7') are minimal ST-separators.

Reduction Rule Max Min st-sep 3. If there exists v € N(95) (resp. v € N(T')), such that
every path from v to any vertex of T" (resp. S) intersects N(S) (resp. N(T')), or there is no
path from v to any vertex of T (resp. S), then update S := S U{v} (resp. T :=T U {v}).

Lemma 10.5.4. Reduction Rule[3]is safe.

Proof. From Lemma no minimal ST-separator, that is disjoint from S U T, contains
v. Since v € N(S) (resp. v € N(T')), for any minimal ST-separator Z, v is reachable from S
in G—Z,since ZNS =0 (resp. ZNT = (). Thus, Z is also a minimal separator between
SU{v}and T. O

Lemma 10.5.5. When Reduction Rule is no longer applicable, N(S) (resp. N(T)) is a

manimal ST-separator.

Proof. First note that N(S) (resp. N(T')) is an ST-separator in G which is disjoint from
SUT, since N(S)NT = 0 because E(S,T) = 0.

For the sake of contradiction, say N (S) is not a minimal ST-separator in G. In particular,
there exists v € N(5) such that N(S)\ {v} is also an ST-separator. Since Reduction Rule
is no longer applicable, there exists a path from v to a vertex of T, say ¢/, which has no
other vertex of N(S). Such a path together with an edge from v to a vertex of S, gives an
ST-path, which intersects N(S) only at v. ]

From Lemma [10.5.5] the safeness of Reduction Rule is immediate.
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Reduction Rule Max Min st-sep 4. If |[N(S)| > k or |N(T)| > k then report a yes

instance.

Reduction Rule Max Min st-sep 5. If N(S)\ N(T) = (0 or N(T)\ N(S) = 0, then

report a no instance.

Lemma 10.5.6. Reduction Rule@ 15 safe.

Proof. Suppose N(S)\ N(T) = (. The other case is symmetric. Then any ST-path uses
only the vertices of SUT U (N(S)NN(T)). In this case there is a unique ST-separator in G
which is disjoint from S UT. This separator is N(S) N N(T). Since Reduction Rule [2|is no
longer applicable, |[N(S) N N(T')| < k. Thus G has no ST-separator of size at least k. [

Branching Rule 1. If|S| < |T| (resp. |T| < |S|) and N(S)\N(T') # 0 (resp. N(T)\N(S) #
), then for each vertex x € N(S)\ N(T) (resp. z € N(T)\ N(S)), we recursively solve the
instance (G,S U{z}, T, k,q) (resp. (G,S, T U{z},k,q)).

First observe that the new instances created in this branching rule are all valid, that is,
the sets S U {x}, T (respectively S, T'U {x}) satisfy the desired properties: the two sets
SU{z} and T (resp. S and T'U{x}) are disjoint, G[SU{z}] (resp. G[T'U{x}]) is connected
and Eq(SU{z},T) =0 (resp. Eg(S,T U{x})) because z € N(S)\ N(T).

Lemma 10.5.7. Branching Rule 18 exhaustive, that s, G has a minimal ST-separator
of size at least k which is disjoint from S UT if and only if there exists x € N(S)\ N(T)
(resp. x € N(T)\ N(S)) such that G has a minimal separator of size at least k between
SU{z} and T (resp. S and T U {zx}), which is disjoint from SUT U {z}.

Proof. Assume that |S| < |T|. The other case is symmetric. Since Reduction Rule [4]is no
longer applicable, |[N(S)| < k. Since Reduction Rule is no longer applicable and because
of Lemma N(S) is a minimal ST-separator. Also because N(S)N N(T') is contained
in every minimal ST-separator in G (from the proof of Lemma , for any minimal
ST-separator in G of size at least k, say Z, which is disjoint from SUT, there exists a vertex
x € N(S)\ N(T) such that x ¢ Z. Since z € N(S), x remains reachable from S in G — Z.

In particular, Z is also a minimal separator between S U {z} and T.
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In the other direction say the instance (G,S U {z}, T, k, ¢q) reports a minimal separator,
say Z, between S U {x} and T of size at least k. Since S U {x} and T satisfy the desired

properties of a valid instance, Z is also a minimal ST-separator in G. m

Proof of Theorem[10.2.]] Initialize an instance I = (G, S, T, k,q) where S = {s} and T =
{t}. Note that u(I) = ¢ — 1. Apply Reduction Rules exhaustively in-order. Without
loss of generality, say |S| < |T'|, the other case is analogous. Since none of the reduction
rules are applicable, 1 < |N(S) \ N(T')| < k. Now apply Branching Rule |1} After every
application of the Branching Rule wu(I'), where I’ is the new instance, strictly decreases
if |S| # |T|. If |S| = |T|, then after every two applications of the Branching Rule (1} the
measure £ decreases by 1. From Reduction Rule[l] if 4 of an instance is at most 0, then we
stop and report a yes instance. The correctness of this algorithm follows from the safeness
of the Reduction Rules[1}5]and Branching Rule[1] We now argue about the running time.

Note that all reduction rules can be applied in polynomial time. Also all reduction
rules, except Reduction Rule|3| is applied only once throughout the algorithm. Reduction
Rule is applied at most 2¢ times (or until both S and T' grow to a size of ¢ each). Thus
only polynomial time is spent on all applications of all reduction rules. The branching rule
branches in at most £ — 1 instances and has depth bounded by 2¢q. Therefore the overall

running time is (k — 1)%7 . n@@). O

10.6 NP-hardness of MAXIMUM MINIMAL OCT

Lemma 10.6.1. MAXIMUM MINIMAL OCT is NP-hard.

Proof. 1t was shown in [81] that MAXIMUM WEIGHT MINIMAL $t-SEPARATOR is NP-hard
on bipartite graphs, even when all vertex weights are identical. This implies that MAXIMUM
MINIMAL st-SEPARATOR is NP-hard on bipartite graphs. We provide a polynomial-time
reduction from the MAXIMUM MINIMAL st-SEPARATOR to the MAXIMUM MINIMAL OCT.
Given an instance I = (G, s,t, k) of the MAXIMUM MINIMAL st-SEPARATOR, we construct
an instance I’ = (G', k' = k) of the MAXIMUM MINIMAL OCT as follows. We assume that

k > 1. We consider two cases based on the bipartition of G:
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Case 1: If s and t are on the same side of the bipartition, we add an edge between s and ¢,

and subdivide it by adding two vertices u and v, creating a new graph G'.

Case 2: If s and t are on opposite sides of the bipartition, we add a subdivided edge between

s and t with one new vertex v/, resulting in G’.
In both cases, the newly added path between s and ¢ in G’ is denoted by P’.
We now prove that the instances I and I’ are equivalent.

In the forward direction, let Z be a minimal st-separator in G of size at least k. We claim
that Z is a minimal oct in G’. Since G is bipartite, any odd cycle in G’ must involve s and
t. Removing Z from G separates s and ¢, meaning G’ — Z contains only the newly added

path P’, ensuring no odd cycles in G’. Thus, Z is an oct in G'.

Next, we show that Z is minimal. For every z € Z, the graph G — (Z \ {z}) contains a
path between s and t. Lets call it P. In Case 1, this path is even-length, and in Case 2, it
is odd-length. In both cases, the graph G’ — (Z \ {z}) contains two vertex-disjoint paths P
nad P’ between s and t of different parities, forming an odd cycle. Therefore, Z is a minimal

oct in G,

In the backward direction, let Z’ be a minimal oct in G’ of size at least k. Since k > 1,
Z' does not include the newly added vertices u,v (in Case 1) or v’ (in Case 2). We claim

that Z’ is a minimal st-separator in G.

If Z' were not an st-separator in G, then there would exist a path between s and ¢ in
G — 7', implying the presence of an odd cycle in G’ — Z’ involving the path P’. Since Z’
is a minimal oct in G, for every z € Z’, the graph G’ — (Z’\ {z}) contains an odd cycle.
Therefore, G’ — (Z'\ {z}) must contain a path P which is a vertex-disjoint st-path from P’.
This implies that G—(Z’\{z}) contains an st-path, proving that Z’ is a minimal st-separator
in G. Thus, I and I’ are equivalent, completing the proof. m

Lemma 10.6.2. Given a graph G and a vertex v € V(G), determining whether there is an

induced odd cycle containing a given vertex is NP-complete.

Proof. We know that given two vertices a and b, determining if a graph contains an induced

path of odd length between a and b is NP-complete [15]. Construct a graph G’ by adding a
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new vertex x to G and making it adjacent to vertices a and b. It is clear that there exists
an induced odd cycle through z in G’ if and only if there is an induced path of odd length
between a and b in G. This finishes the proof. m

Note that the Lemma [10.6.2| also shows that given a vertex x € V(G), it is NP-hard to

determine if there exists a minimal odd cycle transversal containing x.

10.7 MAXIMUM MINIMAL OCT parameterized by solution

size

In this section, we prove Theorem|10.2.2

Throughout this section, we call a set of vertices of G whose deletion results in a bipartite
graph, an oct of G. To prove Theorem |10.2.2] we first show that the problem is CMSO

definable (Lemma[10.7.1). Using Proposition|10.2.3| one can reduce to solving this problem
on (q,2k)-unbreakable graphs. On (g, 2k)-unbreakable graphs, we then list and prove two

sufficient conditions (Lemmas|10.7.5|and [10.7.4)) which always imply a yes instance (in fact

the first one implies a yes instance even when the input graph is not (g, 2k)-unbreakable).
We also make an observation about irrelevant vertices that can be deleted without changing
the solution of the instance (Observation . Even though checking whether any one
of these sufficient conditions hold or finding these irrelevant vertices, may not be efficient,
nonetheless we design an FPT algorithm that correctly concludes that at least one of the
sufficient conditions is met, or outputs an irrelevant vertex, whenever the number of vertices
in the graph is strictly more than a number with is a function of ¢ and & (Theorem .
In the case when an irrelevant vertex is outputted, deleting them reduces the size of the
graph but the resulting graph may not be (¢, 2k)-unbreakable. In this case, we start from
the beginning and solve the problem from scratch (on general graphs). If none of the above
hold, then the number of vertices in the graph is bounded, and we can solve the problem

using brute-force.

Lemma 10.7.1. MAXIMUM MINIMAL OCT is CMSO-definable by a formula of length O(k).

Proof of Lemma|10.7.1, Let (G,k) be an instance of MAXIMUM MINIMAL OCT. Then
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(G, k) is a yes instance if there exists Z C V(G) of size at least k such that G — Z is
bipartite and for each v € Z, G — (Z \ {v}) is not bipartite.

Alternately, let Z C V(G) such that Z contains k distinct vertices vy, . .., vk, such that
G — Z is bipartite and for each i € [k], G — (Z \ {v}) is not bipartite. Observe that if such
a set Z exists, it may not be a minimal oct of GG, but it definitely contains a minimal oct of
size at least k. In fact, (G, k) is a yes instance if and only if such a set Z exists. We phrase
this description of Z as the CMSO formula 1) as defined below.

=37 CV(GQ) (Elvl,vg,...,vkeZ ( /\ vﬁévj)
1<i<j<k

A bipartite(V(G) \ Z)

A </\ —bipartite(V(G) \ (Z \ {%})) )

where bipartite(11) is a CMSO sentence given below, which checks whether the graph
induced by the vertices in W is bipartite.

bipartite(W) =3X C W,3Y C W

((XﬂYzQ))/\(XUY:W)

AVu,v e W (E(u,v) = (ue X <= vEY))).

It is clear that the size of the above formula ¢ depends linearly on k. O

Because of Lemma [10.7.1|we can invoke Proposition [10.2.3] We invoke Proposition [10.2.3
with ¢ = 2k. Let ¢ be the s from this proposition that corresponds to this choice of c. We

conclude that to prove Theorem [10.2.2]it is enough to prove Theorem [10.2.5

Theorem (Theorem|10.2.5)). For any positive integers q, k > 1 MAXIMUM MINIMAL OCT
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on (q, k)-unbreakable graphs is FPT parameterized by q + k.

We prove Theorem [10.2.5|in Section [10.7.1] Next we define a certificate for minimality
of oct for a vertex set V. The existence of such certificates guarantees the existence of a

minimal oct which contains V.

Definition 10.7.1. Given a graph G and a set of vertices V' C V(G), we say that an induced
subgraph G' of G is a certificate for the oct-minimality of V', if G' is bipartite and for every
ve V', GIV(G) U{v}] contains an odd cycle.

Lemma 10.7.2. Given a graph G and a set of vertices V', if there is a certificate for the

oct-minimality of V' then there exists a minimal oct of G that contains all the vertices in V.

Proof. Let G’ be a certificate for minimality of V’. First observe that V'NV(G’) = () because
G’ is bipartite, but G[V(G") U{v}], for any v € V', contains an odd cycle. We now construct
a minimal oct of G, say Z, iteratively as follows. Initialize Z = (). Fix an arbitrary ordering
of the vertices in V(G) \ V(G’) (note that V' C V(G) \ V(G')). Traverse the vertices of
V(G) \ V(G') in this order. For any vertex v € V(G) \ V(G') in the chosen order:

e if G[V(G') U {v}] is bipartite, update G’ := G[V(G') U {v}], that is add v to G,

otherwise

o G[V(G") U{v}] contains an odd cycle, in which case add v to the set Z.

When all the vertices in V(G) \ V(G’) have been processed as stated above, then the set Z
is a minimal oct of G. Moreover, one can observe that if we start with G’ which a certificate
for minimality of V’ then V' C Z. O

10.7.1 MAXiMUM MINIMAL OCT on (¢, 2k)-unbreakable graphs

The goal of this section is to prove Theorem|10.2.5| Let (G, k) be an instance of MAXMIN
OCT. A vertex v € V(G) is called irrelevant if (G, k) is equivalent to (G — v, k). To prove

Theorem (10.2.5]it is enough to prove Theorem [10.7.3

Theorem 10.7.3. For positive integers q,k > 1, given as input a graph G which is (g, 2k)-
unbreakable on at least (2q + 2)*(2q + 2)!(k — 1)?772 + 1 wvertices, there exists an algorithm
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that runs in time (qgk)®@ -n°W and either returns a minimal oct of G of size at least k or,

outputs an irrelevant vertex v.

To see the proof of Theorem [10.2.5]assuming Theorem [10.7.3] observe that if the number
of vertices is at most (2¢ + 2)%(2¢ + 2)!(k — 1)?¢™2, then the problem can be solved using
brute-force. Otherwise, the algorithm of Theorem either reports a yes instance, or
finds an irrelevant vertex v, in which case, delete v from the graph and solve the problem

on G — v (which is not necessarily (g, 2k)-unbreakable). The rest of the section is devoted

to the proof of Theorem [10.7.3

Irrelevant vertices.

Observation 10.7.1. If v does not participate in any induced odd cycle, then v is irrelevant.

Proof. For the forward direction, let Z be a minimal oct of G of size at least k. Then G — Z
is bipartite. Additionally, for any z € Z, the graph G — (Z \ {z}) contains an odd cycle
through z, implying the existence of an induced odd cycle C, containing z. As the cycle is
an induced odd cycle, we know that the vertex v does not lie on this cycle. We will show
that Z is also a minimal oct in G —v. Clearly, G — (ZU{v}) is a bipartite graph. Therefore,
Z is an oct of G —wv. For any vertex z € Z, there is an induced odd cycle C, in G —(ZU{v}).

Therefore, Z is also a minimal odd cycle traversal of G — v. m

Note that we cannot explicitly design a (polynomial-time) reduction rule based on the
above observation, because determining whether there is an induced odd cycle containing a
given vertex is NP-complete (see Section [10.6).

Sufficient condition 1 [Long induced odd cycle in G].

Lemma 10.7.4. For any positive integers q,k, if G is (q,2k)-unbreakable and there exists
an induced odd cycle in G of length at least 2q + 2, then G has a minimal oct of size at least
k.

Proof. Let C' be an induced odd cycle of length at least 2¢ + 2 in G. Let z,y € V(C) be
arbitrarily chosen vertices such that C'\ {z,y} contains exactly two paths S and T" each of

length at least ¢ each. Moreover since C' is an odd cycle one of these two paths is odd and
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the other is even. Without loss of generality, we can assume that S is a path of even length

and 7' is a path of odd length. Let Z, := {z} and Z, := {y}.

Below we define a procedure that iteratively grows the sets in (S, T, Z,, Z,) while main-

taining the following invaraints.

e The sets S, T and Z, U Z, are pairwise disjoint.
e (G[S] is connected, bipartite and |S| > q.
e ([T is connected, bipartite and |T'| > q.

e G[SUT U{y}| is a certificate for the oct-minimality for Z, (and in particular, G[S U
T U {y}] is bipartite).

o G[SUT U{z}] is a certificate for the oct-minimality for Z, (and in particular, G[S U
T U {x}] is bipartite).

e Nz)NS#0, N(x)NT # 0, N(y)NS # 0 and N(y)NT # 0.

Observe that the starting sets (5,7, Z,, Z,) defined earlier satisfy these invariant. Since
the iterative procedure grows these sets, the last property always hold. Also G[SUT U{z} U
{y}] contains the odd cycle C. The idea is to grow these sets until Z, or Z, has size at least
k. If this happens then we can use Lemma to conclude that G has a minimal oct of

size at least k.

Since G is (q,2k)-unbreakable and |S|,|T'| > ¢, we have that every ST-separator in G
has size at least 2k + 1. In particular, |N(S) U N(T')| > 2k + 1. Thus, if we guarantee that
(N(S)UN(T)) € (Z, U Z,), then |Z, U Z,| > 2k + 1. Hence either |Z,| > k or |Z,| > k.

Towards this we grow the sets in (5,7, Z,;, Z,) as follows. Let us call the vertices in
SUTUZ,U Z, as marked.

Claim 10.7.1. Let v € N(S) N N(T) be an unmarked vertex. Either G[SUT U{y,v}] or
G[SUT U {x,v}| contains an odd cycle.

Proof. Since both G[S] and G[T] are connected bipartite graphs, there exists a unique bi-
partition, say S = AgU Bg and T' = Ay U By of S and T respectively. Recall that = has
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neighbours in both the sets S and T'. Since the graph G[S U {z}] is bipartite, without loss
of generality, assume that (N(xz) N S) C Bg. Since G[T' U {x}] is also bipartite, without
loss of generality assume that N(z) N7 C Byp. Since the graph G[S U T U {z}] is con-
nected and bipartite, we know that there is an unique bipartition of G[S U T U {z}] which
is GISUT U {z}] = ((As U Ap U {z}) U (Bs U Br)).

Now, let us focus on the connected graph G[SUT U{y}|. Because G[SU{y}] is bipartite,
without loss of generality, we can assume that (N(y) N S) C Beg.

We now show that (N(y) N'T) C Ar. For the sake of contradiction, assume that this is
not the case. This implies that there are two possibilities. If y has neighbours in both the
sets Ar and By then it leads to a contradiction as G[T'U {y}| is bipartite. If the neighbours
of y are contained in the set By then it leads to a contradiction as it would imply that
G[SUT U {x,y}] is bipartite.

Therefore, we get an unique bipartition G[SUT U{y}] = ((As U By U {y}) U (Bs U Ar))
of G[SUT U{y}]. As the vertex v has neighbours in both the sets S and T, there are four

possibilities.

If v has a neighbour in Ag and Az then G[SUT U {y,v} contains an odd cycle.

If v has a neighbour in Ag and By then G[SUT U {z,v} contains an odd cycle.

If v has a neighbour in Ay and Bg then G[SUT U {z,v} contains an odd cycle.

If v has a neighbour in Ay and By then G[S UT U {y,v} contains an odd cycle.
This finishes the proof of the claim. >

Case 1: v € N(S) N N(T). From Lemma[10.7.1] either G[SUT U{z, v}] or G[SUTU{y,v}]
or both contain an odd cycle. If G[S U T U {x,v}] contain an odd cycle, then update
Z, = Z,U{v}. If GISUT U{y,v}] contain an odd cycle, then update Z, := Z, U {v}.
If both are true then update Z, := Z, U{v} and Z, := Z, U {v}. The sets S, T remain

the same. Observe that in either case, the updated sets satisfy all the invariants.
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Case 2: v € N(S)\ N(T). In this case, if G[SU{v}] is bipartite, then update S := SU{v}.
The other sets T, Z,, Z, remain the same. Note that the updated sets (in particular
S) maintains the invariant. Otherwise G[S U {v}]| contain an odd cycle. In this case
update Z, := Z, U {v} and Z, := Z, U {v}.

Case 3: v € N(T) \ N(S). This case is symmetric to Case 2.

We repeat the above process until we have sets (5,7, Z,, Z,) such that all vertices in
N(S)UN(T) are marked. In particular, in this case (N(S)UN(T)) C (Z,U Z,). As argued
earlier, this implies either |Z,| > k or |Z,| > k. In both cases, we report that G' has a
minimal oct of size at least k from Lemma [10.7.2] O

Sufficient condition 2 [Large family of short induced odd cycles].

Lemma 10.7.5. Let (G,k) be an instance of MAXIMUM MINIMAL OCT. Let d be any
positive integer. If F is a family containing distinct induced odd cycles of G of length at
most d and |F| > d(d!)(k — 1)¢ then G has a minimal oct of size at least k.

Proof. From the Sunflower Lemma (Theorem [10.1.1), we can conclude that there exist at
least k induced odd cycles {F}, Fy, ..., F,} C F such that V(F;)NV (F;) =Y foralli, j € [k].
As the cycles in F are induced odd cycles, the graph induced by the set of vertices in Y must
be bipartite. Note that Y could possibly be empty. We claim that G’ has a minimal odd
cycle transversal of size at least k. Such a minimal odd cycle transversal can be obtained
by the greedy algorithm described in the proof of Lemma We start with the induced
subgraph G[Y]. Clearly, any minimal odd cycle transversal obtained by this algorithm will
contain at least one vertex from V' (F;)\ Y for each i € [k]. Since the sets V(F;) \ Y for each

i € [k] are disjoint, a minimal odd cycle transversal must have a size of at least k. ]

The combination lemma.

Lemma 10.7.6. Given a graph G, a vertex x € V(G) and positive integers d, k, there is an

algorithm that runs in (kd)o(d) -nPW time, and correctly outputs one of the following:

1. an induced odd cycle containing x,
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2. an induced odd cycle of length at least d,

3. a family F of distinct induced odd cycles, each of length at most d — 1, such that
|\ F| >d-d-(k—1),

4. a determination that there is no induced odd cycle containing x in G.

Proof. Suppose G contains an induced odd cycle containing x. Let C, be one such cycle. We
design an iterative algorithm that maintains a pair (G’, F), where G’ is an induced subgraph
of G and F is a family of induced odd cycles of length at most d —1 in GG, with the following
additional properties. The graph G’ is guaranteed to contain the cycle C,, if C, existed in
the first place in GG, and every induced odd cycle in G’ is distinct from any cycle in the family
F.

Initialize G’ := G and F = (). In each iteration, the algorithm finds an arbitrary induced

odd cycle of GG, say F', in polynomial time, if it exists. The following cases can now arise.

1. The algorithm fails to find the cycle F'. That is G’ has no induced odd cycle. In this
case, report that G has no induced odd cycle passing through x.

This is correct because if G had such an induced odd cycle passing through z, then
C, exists in G and by the invariants of the algorithm C, also exists in G’, which is a

candidate for the cycle F'.
2. If F contains x, then return F' as the induced odd cycle containing x.

3. If the length of F' is at least d, then return F’ as an induced odd cycle of length at least
din G.

4. Otherwise, F' exists but does not contain x and has a length of at most d — 1.

By the invariants of the pair (G’, F), F is distinct from all the cycles in F. Update F
by adding F to it. Then the updated F satisfies the required invariants.

To update G’ proceed as follows. Guess the intersection of V(F') with V(C,) (in G').
Let this be F’. The number of guesses is bounded by 2/¥1 < 24-1. Since F does not
contain z, F' is not equal to C,. Hence, F'\ F" is non-empty. Update G' := G'— (F'\ I").
Observe that the updated G’ contains C,, if the old GG, contained it. Also every induced
odd cycle in the updated G’ is distinct from F' (that has been newly added to F) because
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a non-empty subset of V(F') (that is V(F') \ V(F”)) has been deleted in the updated
G

If the first, second or third condition mentioned above does not apply in each of the first
d-d! - (k—1)? iterations, then at the end of the i-th iteration where i = d-d!- (k — 1)4,
|F| =d-d!'-(k—1)% In this case, we return F as a large family of induced odd cycles of
length at most d — 1 of G.

This finishes the description and correctness of the algorithm. For the running time
observe that in each iteration, the algorithm runs in polynomial time to find ' and check
the first three conditions. The fourth condition in each iteration makes 297! guesses and

the number of iterations is at most d - d! - (k — 1)?. Therefore the overall running time is
(kd)©@ . nO0), O

Proof of Theorem|10.7.5 The algorithm for Theorem|10.7.3|proceeds as follows. Recall that
G is a (g, k)-unbreakable graph. For each z € V(G), run the algorithm of Lemma|10.7.6[on
input (G, x,2q + 2, k).

If for some x € V(G), the algorithm returns an induced odd cycle of length at least
2q + 2 or a family F of distinct induced odd cycles of length at most 2¢g + 1 such that
|F| > (2¢ + 2)(2q + 2)!(k — 1)?9%2 then we return a yes instance due to Lemma
or respectively. If the algorithm returns that there is no induced odd cycle containing
z in G then we can delete x from G and solve the problem on the reduced graph. This is
safe because of Observation Finally, if the algorithm returns an induced odd cycle
containing x of length at least 2¢q 4+ 2, then again report that it is a yes instance because of

Lemma

If none of the above conditions hold, then for each x € V(G), the algorithm of Lemma|10.7.6
returns an induced odd cycle containing x, say C,, of length at most 2¢ + 1. Note that the

cycles returned for each x € V(G) in this case may not be distinct.

The following claim shows that if the number of vertices in G is large (and there is an
induced odd cycle of small length for each vertex of the graph), then there exists a large
family containing distinct induced odd cycles of small length in G, in which case we can

report a yes instance by Lemma [10.7.5
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Claim 10.7.2. If the number of vertices in G is at least (2q + 2)*(2q + 2)!(k — 1)%+2 4 1,

then G contains a minimal oct of size at least k.

Proof. We will construct a F of distinct induced odd cycles in G of length at most 2¢+1. For
every vertex x € V(G), we take a cycle C,.. Unless the cycle C, is already in F, we will update
F = FU{C,}. As the length of cycles in C, returned by the above algorithm is bounded
by 2q + 1, if the number of vertices in G is more than (2¢ + 1)(2q + 2)(2¢ + 2)!(k — 1)272
then [F| > (2¢ + 2)(2¢ + 2)!(k — 1)?7*2. Due to Lemmal[10.7.5] we return a yes instance. >

This finishes the proof of Theorem |10.7.3

10.8 Closing Remarks and Future Directions

In this work, we established that both MAXIMUM MINIMAL st-SEPARATOR and MAXIMUM
MINIMAL ODD CYCLE TRANSVERSAL (OCT) are fixed-parameter tractable parameter-
ized by the solution size. Instead of using treewidth-based win-win approaches, we design
FPT algorithms for highly unbreakable graphs for both these problems. While we have
demonstrated the FPT nature of these problems, the challenge of developing efficient FPT
algorithms remains open. Additionally, the edge-deletion version of the MAXIMUM MINI-
MAL OCT can be shown to be FPT using similar techniques, but with much simpler ideas.
But designing an faster/explicit FPT algorithm even for this version remains an interesting
direction for future research. Finally, the parameterized complexity of the weighted version
of MAXIMUM MINIMAL st-SEPARATOR and MAXIMUM MINIMAL WEIGHT OCT remain
open as very large weights cause problems while formulating the problem in CMSO, in order

to reduce it to the unbreakable case.
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Chapter 11

Conclusions and Open Problems

This thesis explores the parameterized complexity of several graph problems, including
HARMLESS SET, DEFENSIVE ALLIANCE, OFFENSIVE ALLIANCE, LOCALLY AND GLOB-
ALLY MINIMAL DEFENSIVE ALLIANCES, F-FREE EDGE DELETION, T},.;-FREE EDGE
DELETION, and MAXMIN SEPARATION PROBLEMS. These problems are not only of theo-
retical interest but also have practical relevance in areas like social networks, epidemiology,

and optimization.

A key highlight of this thesis is the resolution of several open problems mentioned in the

literature. Specifically:

e We resolved an open question posed by Enright and Meeks [44] by proving that the
T+1-FREE EDGE DELETION problem is W[1|-hard when parameterized by treewidth.

e We addressed an open question raised by Hanaka et al. [8I] regarding the fixed-
parameter tractability of the MAXIMUM MINIMAL st-SEPARATOR problem, demon-

strating that it is FPT when parameterized by solution size.

e We resolved an open problem posed by Bazgan et al. [10] regarding the parameterized
complexity of HARMLESS SET parameterized by treewidth, showing that it is W][1]-
hard.

e We answered an open question by Bernhard Bliem and Stefan Woltran [16] concerning

the complexity of the OFFENSIVE ALLIANCE problem parameterized by treewidth,
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proving its W]|1]-hardness.

This thesis presents new FPT algorithms, complexity classifications, and kernelization

results. For example:

e We provided FPT algorithms for HARMLESS SET under parameters such as vertex

integrity, twin cover and neighborhood diversity.

e We developed kernelization techniques for LOCALLY MINIMAL DEFENSIVE ALLIANCE

on specific graph classes and subexponential algorithm on planar graphs.

e We demonstrated the W|[1|-hardness of problems like HARMLESS SET, DEFENSIVE
ALLIANCE and OFFENSIVE ALLIANCE under restrictive structural parameters such as
treedepth, feedback vertex set, pathwidth etc. showcasing the inherent complexity of

these problems.

This thesis not only advances the theoretical understanding of these problems but also
lays the groundwork for future research. Potential directions include designing faster algo-
rithms for MAXMIN SEPARATION problems, addressing the weighted versions of HARMLESS
SET and considering MAXIMUM MINIMAL st-SEPARATOR on directed graphs, and exploring
complexity of 7,.1-FREE EDGE DELETION parameterized by restrictive structural parame-

ters along with solution size as a parameter.

In summary, this thesis significantly contributes to the landscape of parameterized com-
plexity, offering a deeper understanding of fundamental graph problems, resolving long-
standing open questions, and providing new tools and techniques for tackling challenging

computational problems.
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